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Abstract

Algorithms is a broad, rich, and fast-growing field. For the latter half of last
century, many branches of algorithms have emerged and grown in popularity, and
many different techniques have been invented to solve the central problems in each
area. Some of these techniques, such as the push-relabel algorithm for maximum
flow, are specially designed to solve a single problem. Other techniques, such as
the multiplicative weights update method, are more general and applicable to a
wide range of problems. And others, such as dynamic programming, divide and
conquer, and linear programming relaxation and rounding, are so fundamental
that they have not only pervaded every branch of algorithms, but have ultimately
reshaped the way we approach algorithm design.

This thesis is devoted to studying two more modern algorithmic techniques,
namely preconditioning and locality, which were pioneered by Spielman and
Teng [100] in their ground-breaking work on Laplacian system solvers and have
seen countless new applications in the past decade. In this thesis, I successfully
apply preconditioning and locality to resolve fundamental open problems from
a wide array of algorithmic subfields, from fast, sequential algorithms to deter-
ministic algorithms to parallel algorithms, thereby demonstrating the power and
versatility of the two techniques. Taking one step further, I make my case that
preconditioning and locality are more than just powerful tools with countless
applications: they are new, fundamental ways of thinking about algorithms that
have the potential to revolutionize algorithm design just like dynamic program-
ming and divide and conquer had done in the past.
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Chapter 1

Introduction

Algorithms is a broad, rich, and fast-growing field. For the latter half of last century,
many branches of algorithms have emerged and grown in popularity, and many different
techniques have been invented to solve the central problems in each area. Some of these
techniques, such as the push-relabel algorithm for maximum flow, are specially designed to
solve a single problem. Other techniques, such as the multiplicative weights update method,
are more general and applicable to a wide range of problems. And others, such as dynamic
programming, divide and conquer, and linear programming relaxation and rounding, are
so fundamental that they have not only pervaded every branch of algorithms, but have
ultimately reshaped the way we approach algorithm design.

This thesis is devoted to studying two more modern algorithmic techniques, namely
preconditioning and locality, which were pioneered by Spielman and Teng [I00] in their
ground-breaking work on Laplacian system solvers and have seen countless new applications
in the past decade. In this thesis, we successfully apply preconditioning and locality to resolve
fundamental open problems from a wide array of algorithmic subfields, from fast, sequential
algorithms to deterministic algorithms to parallel algorithms, thereby demonstrating the
power and versatility of the two techniques. Taking one step further, we make our case that
preconditioning and locality are more than just powerful tools with countless applications:
they are new, fundamental ways of thinking about algorithms that have the potential to
revolutionize algorithm design just like dynamic programming and divide and conquer had
done in the past.

In this introductory section, we first introduce the two techniques, preconditioning and
locality, and provide the relevant history and background. Along the way, we illustrate how
both techniques can be applied to the classic minimum cut problem on a graph, deriving
surprisingly simple and fast algorithms for various settings of the problem.
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Figure 1.1: Both graphs above are composed of two cliques connected by a single (red) edge,
the mincut of the graph. The green regions of each graph mark the area a local algorithm
needs to explore, starting from the seed vertex s, before it detects the red edge and certifies
it as a cut. The instance on the left is more amenable to a local algorithm than the one on
the right.

1.1 Locality: Unbalanced vs. Balanced

In the modern, digital era, data sets have become so large that many algorithms cannot
afford to even read in the whole input. In other words, even linear-time algorithms are often
too slow in practice. This dilemma motivates the concept of locality in algorithm design: a
local algorithm is one that only reads in data “local” to a seed location in the input. When the
input is a massive graph, a local algorithm may only explore a small neighborhood around
the seed vertex before outputting the solution. This is the approach taken by many local
graph algorithms with theoretical guarantees, most notably the PageRank Nibble algorithm
for computing a cut of small conductance around a seed vertex of a graph [8, [100].

Of course, for such an algorithm to be possible, the solution to the problem must also be
local to the seed vertex. For illustration, consider the task of finding the (global) minimum
cut of the graph, defined as the smallest set of edges whose deletion disconnects the graph.
Consider the two graphs in Figure [I.1} both are comprised of two cliques attached by a single
edge (marked red), and our task is to locate the red edge, which is a mincut of size 1. From
the seed vertex s, a local algorithm can explore the clique containing s (highlighted in green)
and then discover that the only edge neighboring that clique is the single red edge. This is
enough information to certify that the red edge indeed forms a cut in the graph, at which
point the algorithm can stop and output that cut. If the clique containing s is much smaller
than the entire graph (Figure [L.1] left), then this algorithm avoids reading in most of the
graph, which saves a lot of computation time. On the other hand, if the clique containing s
occupies a large fraction of the entire graph (Figure , right), then the local algorithm still
has to look at most of the graph. In other words, not every instance is amenable to a local
algorithm: the instance on the left of Figure is more “local” than the one on the right.

In this thesis, we advance beyond the concept of local algorithms and study locality as
a fundamental principle in algorithm design. What is so special about locality that enables
us to obtain faster algorithms? More precisely, suppose we have a problem instance where

8



Figure 1.2: Instances of Steiner mincut where the terminal vertices are marked by dots.
The instance to the right satisfies the locality assumption: the Steiner mincut (marked in
red) separates exactly one terminal from the rest. By sampling terminals from the instance
on the left at the correct sampling probability, we can create a new instance that satisfies
the locality assumption (like the one on the right).

the target solution is local to a particular location of the input (possibly unknown to us).
That is, we have a problem instance like the one to the left of Figure [I.1] not the one to the
right. How can we solve the instance faster by exploiting this additional locality assumption,
and what new techniques can we develop along the way? Lastly, since our goal is to design
algorithms that work on all instances, not just local ones, we also need to remove the locality
assumption eventually.

Application: Steiner mincut. We now highlight our locality-based approach to the
mincut problem, which illustrates many of our techniques in a remarkably simple setting.
We actually consider a more general problem called the global Steiner mincut, where we are
given both an undirected graph and a subset of vertices called the terminals, and we want
to find the minimum-weight set of edges whose removal disconnects at least two terminals
from each other (see Figure [1.2). The (global) mincut is simply the Steiner mincut when
every vertex is a terminal.

To approach this problem from a locality perspective, we first specify what it means to
be local in our problem setting. We define a Steiner mincut to be local if it separates ezactly
one terminal from the rest, or in other words, the cut is “local” to that particular terminal
(which is unknown to us). In Figure[1.2] the instance on the left does not have a local Steiner
mincut, but the graph on the right does.

Given this locality assumption, we only need to look at cuts that separate one terminal
from the others. That is, the task reduces to finding, for each terminal, the minimum number
of edges that separate that terminal from the rest. We call this problem the minimum
isolating cuts and design a fast and simple algorithm for it which is covered in Chapter [2|
This solves the Steiner mincut problem under this locality assumption.

Finally, removing the locality assumption turns out to be miraculously easy: we simply
sample a subset of terminals and declare the sample as the new set of terminals. By trying
enough times at various sampling probabilities, we can ensure with high probability that for



some sample, the Steiner mincut has exactly one sampled terminal on one side (see Figure ,
right). We can therefore solve Steiner mincut with the additional locality assumption on this
sampled terminal set and obtain the Steiner mincut on the original instance.

This simple algorithm is covered entirely in the short Chapter [ and Section [3.2] and yet
it was the first nontrivial Steiner mincut algorithm on weighted graphs. Looking back, we
believe that the key insight that eluded researchers in the past was viewing the problem from
a locality perspective. Once the locality assumption is established, the remaining pieces fall
together almost seamlessly.

1.1.1 Minimum Isolating Cuts and Applications

The aforementioned minimum isolating cuts problem turns out to play a central role in many
of our locality-based algorithms. We recall the problem definition: the input is a graph with
a subset of vertices as terminals, and the goal is to compute, for each terminal, the minimum
cut that separates that terminal from the rest of the terminals. In other words, we want to
compute a mincut “local” to each terminal.

Clearly, minimum isolating cuts can be trivially solved using |R| calls to (s,t)-mincut.
What is surprising is that we can do much better: we design a simple algorithm requiring
only O(log|R|) calls to (s,¢)-mincut. In other words, minimum isolating cuts and (s,?)-
mincut have the same time complexity up to this logarithmic factor. We call this result the
isolating cuts lemma, stated and proved in Chapter 2]

Global Steiner connectivity (Chapter . As mentioned before, one immediate con-
sequence of the isolating cuts lemma is a randomized algorithm for global Steiner mincut
problem in roughly (s, ¢)-mincut time, already the fastest known for general, weighted graphs.
The simple algorithm and analysis is presented in Chapter [3]

Theorem: Randomized Steiner mincut (see Theorem 3.2.1)

There is a randomized Steiner mincut algorithm that runs in O(log® n) many calls to
(s,t)-mincut.

We then derandomize our algorithm, which requires technical tools from derandomiza-
tion as well as expander decompositions. Naturally, the running time also becomes larger,
although for the current running time of (s, t)-mincut, the additional overhead is only poly-
logarithmic. Even for the deterministic global mincut problem, this was the first improve-
ment over the O(mn) time algorithm of Hao and Orlin [48]. We present the deterministic
Steiner mincut algorithm in Section [3.3]

10



Theorem: Deterministic Steiner mincut (see Theorem 3.3.1)

For any constant € > 0, there is a randomized Steiner mincut algorithm that runs in
polylog(n) many calls to (s, t)-mincut, plus O(m!*) additional time.

Gomory-Hu tree (Chapter 4]). In Chapter , we use the isolating cuts lemma to develop
locality-based algorithms that compute the Gomory-Hu tree, a classic data structure that
encodes all pairwise (s,?)-mincuts of a graph. In particular, given the Gomory-Hu tree, we
can answer any (s,t)-mincut queries in constant time per query.

Gomory and Hu [44] showed that a Gomory-Hu tree can be computed using n — 1 many
(s,t)-mincut computations, and for general, weighted graphs, this bound has yet to be
improved sixty years later. While we were unable to break this barrier, we managed to
reduce the Gomory-Hu tree problem to a seemingly much simpler problem, which we call
single-source mincut verification: given a source vertex s and (s,t)-mincut overestimates

A(s,t) for all other vertices ¢, determine which estimates A(s,t) equal the true (s, )-mincut
values.

Theorem: Gomory-Hu tree from single-source mincut verification (see Theorem 4.2.3)

There is a randomized Gomory-Hu tree algorithm that makes calls to single-source
mincut verification on graphs with a total of O(n) vertices and O(m) edges, and runs
for max-flow time outside of these calls.

Note that this problem is even simpler than single-source mincut, where we are given the
source vertex s and need to compute the (s, v)-mincut for each v € V'\ s. Unfortunately, we
do not know how to solve even the verification problem faster than computing a separate
(s,v)-mincut for each v € V \ s. Nevertheless, as a simpler, seemingly more tractable
problem, single-source mincut verification may prove the key to obtaining faster Gomory-Hu
tree algorithms in the future.

Approximate Gomory-Hu tree (Chapter 4)). If we relax the problem to computing an
approximate Gomory-Hu tree, then we can indeed obtain faster algorithms. Our main result
in Section [4.7is an algorithm for approximate Gomory-Hu tree that makes polylogarithmic
calls to exact max-flow. This algorithm essentially follows from the fact that the reduction to
single-source mincut verification is robust to approximations, and that approximate single-
source mincut can be computed in faster time. To solve approximate single-source mincut,
we introduce a new problem which we name the cut threshold problem: given a source vertex
and a parameter A called the cut threshold, find all vertices whose pairwise mincut with the
source is at most .

Using the isolating cuts lemma, we show that the cut threshold problem can be solved in
roughly max-flow time. We then reduce approximate single-source mincut to the cut thresh-

11



old problem by trying geometrically increasing values of A. Putting everything together, we
obtain an approximate single-source mincut algorithm in roughly max-flow time, and by the
aforementioned reduction, an approximate Gomory-Hu tree as well.

Theorem: Approximate Gomory-Hu tree (see Theorem 4.2.5)

There is a randomized (14 ¢)-approximate Gomory-Hu tree algorithm that makes calls
to max-flow on graphs with a total of O(n) vertices and O(m) edges.

Directed global mincut (Chapter Last but not least, we study the global mincut
problem in directed graphs in Chapter [5{ and reduce the problem to O(y/n) many max-flow
calls. Using the current best max-flow algorithm [75], our running time becomes O(m+/n +
n?), improving upon the O(mn) bound of Hao and Orlin [48§].

Theorem: Directed mincut (see Theorem 5.3.1)

There is a directed global mincut algorithm that makes O(y/n) max-flow calls.

This is the only result of the locality section that does not rely on the isolating cuts
lemma as a subroutine. Nevertheless, we approach the problem from a locality perspective,
dividing the problem into an unbalanced and a balanced case. For the unbalanced case,
where the optimal cut has a small number of vertices on one side, we use a locality-based
approach similar to the one for deterministic mincut (Chapter @ For the balanced case,
we simply sample vertices s,t at random and compute an (s, t)-mincut, which succeeds with
large enough probability that we can repeat the procedure a small number of times to succeed
w.h.p.

1.2 Preconditioning: Worst Case vs. Average Case

Traditionally, algorithms are studied in the worst-case setting: for an algorithm to be deemed
“fast”, it must run quickly on all instances of the problem. The appeal of worst-case analysis
is its robustness and its emphasis on concrete, universal statements: algorithms must work
well for all inputs. However, designing optimal algorithms for the worst case is difficult,
and it is often easier to consider average-case analysis, where algorithms are designed and
analyzed for well-behaved instances, rather than for worst-case instances. These algorithms
are often faster and cleaner, but they lack the strong universal guarantees of worst-case
algorithms.

Preconditioning is a technique that strives for the best of both worlds: the optimality
and simplicity of average-case analysis and the robustness of the worst-case setting. This is
achieved by transforming, or preconditioning, any input instance to behave like an average-
case instance. In this way, we can focus our attention on well-behaved, non-pathological

12
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Figure 1.3: An expander graph with the global mincut marked in red. The global mincut
is “unbalanced” since one of its sides only has 3 vertices.

instances, and then translate the results back to derive worst-case bounds.

Historically, preconditioning was first developed in the field of numerical linear algebra
to solve linear systems of the form Ax = b. While the matrix A may be difficult to solve
in the worst case, one can, for example, multiply both sides by a matrix M to arrive at the
equivalent (M A)xz = Mb. In this context, preconditioning is the art of choosing the matrix
M so that the new, preconditioned matrix M A is well-behaved. One can then apply iterative
methods which converge in few iterations on a well-conditioned matrix. The first paper to
utilize the term preconditioning is due to Evans [35], though the concept has been employed
to solve linear systems by hand over a century ago [92].

In the context of graph algorithms, preconditioning can be applied in various ways de-
pending on the problem being solved. For distance-related problems, Awerbuch et al. [12]
introduced the concept of low-diameter network decompositions which are now standard in
shortest path and related algorithms. This technique decomposes a general graph into sub-
graphs of small diameter, which often admit faster and simpler distance-based algorithms.
For graph cut and spectral problems, Spielman and Teng popularized the technique of ez-
pander decomposition in their seminal work on solving linear systems on graphs [100]. Here,
general graphs are decomposed into expanders, graphs that exhibit nice cut and spectral
properties. We study both low-diameter and expander decompositions in this thesis.

Application: deterministic mincut. Once again, we illustrate how we can apply the
preconditioning technique to the mincut problem.

Informally, the preconditioning technique reduces general graph instances to the case
when the input graph is an expander. The key property that we exploit is that on an
expander, the global mincut is unbalanced in the locality sense: one of its sides has very few
vertices (see Figure[L.3). This shows how the concepts of preconditioning and locality often
go hand in hand.

How is this guarantee useful for us? While the locality-based Steiner mincut algorithm
works on any graph, it crucially relies on a random sampling procedure, which makes the

13



algorithm inherently randomized. Fortunately, in an expander where the global mincut
is unbalanced, the random sampling procedure can be efficiently derandomized. The de-
randomization technique does not work well on general graphs, however, which is where
preconditioning comes in handy: we precondition the graph to behave like an expander by
computing an expander decomposition of the graph. The final result is a deterministic Steiner
mincut algorithm, discussed in Section

1.2.1 Graph Cut Problems

In this thesis, all problems studied include graphs as part of their input. For graph cut
problems, such as global mincut, the average-case instances that we study are the expander
graphs. This is a natural class of graphs to study in average-case analysis since it includes
the random graphs, e.g., those drawn from the Erdés-Rényi G(n, p) model where each (undi-
rected) edge is independently sampled with probability p.

By assuming that the input graph is an expander, we can appeal to the rich theory
of expanders, including the connection to spectral graph theory established by Cheeger’s
inequality. As for preconditioning the graph, or transforming it into the average case setting,
our main strategy is to compute an expander decomposition of the input graph: we delete
a small fraction of edges so that each connected component of the remaining graph is an
expander. This simple concept, popularized by Spielman and Teng in their seminal paper
on solving Laplacian systems, has proven invaluable in recent breakthroughs in fast graph
algorithms in many areas, from sequential to dynamic to distributed algorithms. Following
an expander decomposition, a common strategy is to first solve the problem separately on
each expander by appealing to average-case analysis. Then, to handle the edges deleted by
the decomposition, we apply recursion on an instance a constant factor smaller in order to
keep the overall running time small. While most preconditioning-based approaches follow
the same general outline, tailoring the method to each individual problem is always the key
challenge and, ultimately, lies at the core of the art of preconditioning.

Deterministic preconditioning (Chapter . Prior to our work, the biggest drawback
to expander decomposition-based algorithms was that the only near-linear time algorithms
to compute an expander decomposition were randomized. In joint work with Chuzhoy,
Gao, Nanongkai, Peng, and Saranurak [27], we develop the first almost-linear time, de-
terministic algorithm for expander decomposition, thus opening the door to deterministic,
preconditioning-based algorithms. Due to the technical complexity of the algorithm, we defer
it to Chapter [§ at the end of the thesis.

Deterministic mincut (Chapter @ We next discuss a new application of deterministic
expander decomposition, namely to the global mincut problem: determine the smallest-
weight set of edges whose removal disconnects the graph.
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A classic result of Karger [55] established a near-linear time randomized algorithm for
global mincut, and Karger famously posed as an open question whether a fast, deterministic
algorithm exists. For almost twenty years, no progress had been made towards even partially
answering this question, until the breakthrough result of Kawarabayashi and Thorup [57]
who achieved a deterministic, near-linear time algorithm on simple, unweighted graphs.
Their key conceptual contribution is a simple but meaningful connection between mincuts
and expanders: on an expander, the global mincut must be unbalanced in the locality sense.
Their work serves as evidence that the global mincut problem should be much easier on an
expander, which suggests a preconditioning-based line of attack.

In recent work [69], we manage to complete the preconditioning approach for the deter-
ministic mincut problem, resolving Karger’s open problem from the 1990s.

Theorem: Deterministic mincut (see Theorem 6.2.1)

14o(1)

There is a deterministic mincut algorithm that runs in m time.

Our strategy is to de-randomize the single randomized component in Karger’s mincut
algorithm, namely the construction of the graph sparsifier by random sampling. We adopt a
preconditioning-based approach, first solving the case when the input graph is an expander,
and then applying expander decomposition to generalize to all graphs. To sparsify an ex-
pander, we exploit the locality of the target mincut as mentioned before (see Figure . In
particular, it suffices to preserve only the unbalanced cuts in the sparsification procedure,
which makes it much easier to derandomize.

For the general case, our strategy is still to preserve a subset of “unbalanced” cuts,
but this time, the notion of unbalanced is defined with respect to a recursive expander
decomposition hierarchy of the graph. We defer the details to our full presentation of the
algorithm in Chapter [6]

1.2.2 Graph Distance Problems

For graph problems involving distances, such as the single-source shortest path problem, the
property we exploit from average-case instances is that they have small aspect ratio, defined
as the ratio of the maximum to minimum distances between distinct vertices of the graph.
Once again, this assumption is consistent with the theory of random graphs in average-case
analysis: for a random graph sampled from the Erdés-Rényi G(n, p) model, where all edges
sampled have unit weight, the aspect ratio is only O(logn).

Parallel shortest path (Chapter [7)). We apply distance-based preconditioning to the
single source shortest path problem in the parallel (PRAM) setting. The parallel shortest
path problem is notoriously difficult, especially in the exact setting, since classic shortest path
algorithms such as Dijkstra’s are inherently sequential. This barrier encouraged researchers
to study the approzrimate single-source shortest path problem instead, for which near-optimal
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parallel algorithms are now known. A classical result of Cohen [28] developed an algorithm
based on the concept of hopsets that runs in O(m!*<) work and polylogarithmic time for
any constant €y > 0, which is optimal up to the m® factor. The natural follow-up question
is whether the work can be improved to m polylog(n), but this question has resisted two
decades of attempts.

In our paper [67|, we tackle this problem from a continuous perspective, using the
preconditioning-based method of Sherman [96, 97]. We reduce the SSSP problem to the
more continuous minimum transshipment problem, also known as uncapacitated minimum
cost flow, and provide (1 + €)-approximate algorithms for both problems in m polylog(n)
work and polylogarithmic time. This improves upon Cohen’s result and achieves the tar-
geted optimality.

Theorem: Parallel SSSP and transshipment (see Theorems 7.1.1 and 7.1.2)

There are parallel (1 + €)-approximate SSSP and transshipment algorithms that run
in O(m) work and polylog(n) time.

Sherman’s preconditioning-based method is based on his key insight that low-diameter
graphs admit a simple transshipment algorithm. To precondition a general graph into low-
diameter graphs, he computes low-diameter decompositions of the graph at varying diameter
scales. For technical reasons, the low-diameter decomposition is performed on an embedding
of the graph into high-dimensional (Euclidean) space, where distances between vertices in
the graph are approximated by distances between their corresponding points in space. We
leave the details to the full presentation in Chapter [7]

1.3 Preliminaries

In this thesis, all graphs are either undirected or directed, and either unweighted or weighted
on the edges. All weighted graphs have positive weights, and unweighted graphs are treated
as weighted graphs with weight 1 on all edges. We allow multiple parallel edges, even on
weighted graphs, but no self-loops unless explicitly stated otherwise.

We first start with standard graph-theoretic notation. For an undirected graph G =
(V, E) and two vertex subsets A, B C V, we denote by E(A, B) the set of all edges with one
endpoint in A and another in B. If G is directed, then Eg (A, B) denotes the set of (directed)
edges from a vertex in A to a vertex in B. For an edge e € E, let w(e) be the weight of that
edge, and for vertices u,v € V, let w(u,v) be the sum of the weights w(e) of all (parallel)
edges e between u and v. For a vertex subset S C V, define 05 := E(S,V \ S). For a subset
F C FE of edges, denote its total weight by w(F). Define the (weighted) degree of a vertex
v eV asw(d({v})), and for a subset S C V, define its volume vol(S) := > _sdeg(v). For
a subset S C V', define N(S) C V as the set of vertices v € V' \ S with a neighbor in S, and
define N[S] = S U N(S). When the graph G is ambiguous, we may add a subscript of G
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in our notation, such as Eg(A, B). When a set in question is a singleton vertex v, we may
write v instead of {v}, such as in F(v, B). Finally, for any graph H, we use V(H) to denote
the set of vertices of H, and E(H) to denote the set of edges of H.

For an undirected graph G = (V| E), we define an (edge) cut to be either (1) the set of
vertices S on one side of the cut, (2) the corresponding bipartition (S, V \ S) of vertices, or
(3) the edges 0S in the cut. We alternate between the three definitions depending on which
one is most convenient for the occasion.
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Chapter 2

Minimum Isolating Cuts

In this chapter, we study the minimum isolating cuts problem introduced in [70]: given a list
of terminals, compute, for each terminal, the mincut separating it from the other terminals.
We present the simple algorithm from [70] that solves this problem in a logarithmic number
of (s,t)-mincut calls.

In just one year after its introduction, the minimum isolating cuts problem has already
seen numerous applications to graph cut algorithms. In Chapters |3[ and [4] of the thesis, we
use the minimum isolating cuts algorithm as a core subroutine in our Steiner mincut and
Gomory-Hu tree algorithms. The minimum isolating cuts has also appeared in problems
ranging from vertex connectivity [73] to submodular function minimization [23], [84], which
are outside the scope of this thesis.

Given the simplicity of the algorithm and its analysis, as well as its importance in recent
developments, it is perhaps miraculous that the minimum isolating cuts problem and algo-
rithm had remained undiscovered for so long. In hindsight, we believe the biggest hurdle to
its discovery was not the technical algorithm itself, but the conceptual realization that the
minimum isolating cuts problem could be so useful in the first place, especially in locality-
based algorithms. In fact, we attribute our discovery of the problem entirely to our locality
perspective: as we will see in Chapter [3] it is exactly the problem to study when solving
Steiner mincut with a locality assumption.

2.1 Background

The minimum isolating cuts problem was first introduced to solve the deterministic mincut
problem [70]. Since its inception, the isolating cuts lemma has been generalized to the
wider setting of symmetric, submodular functions [23] 84], leading to new developments on
submodular function minimization and hypergraph mincut.
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Figure 2.1: The minimum isolating cuts algorithm for |R| = 4. The orange marks the “upper
boundary” of each green isolating cut. They are formed by the min-cut separating {0, 1}
and {2,3} and the min-cut separating {0,2} and {1, 3}.

2.2 The Isolating Cuts Algorithm

We first formally define the minimum isolating cuts problem.

Definition 2.2.1: Minimum isolating cuts

Consider a weighted, undirected graph G = (V, E) and a subset of vertices R C V
where |R| > 2. The minimum isolating cuts for R is a collection of sets {9, : v € R}
such that for each vertex v € R, the set S, satisfies S, N R = {v} and w(9S)) is a
(v, R\ v)-mincut.

We now state the isolating cuts lemma, whose proof occupies the rest of this section.

Lemma 2.2.2: Isolating cuts lemma

Fix a subset R C V of terminals, where |R| > 2. There is an algorithm that computes
the minimum isolating cuts for R using [lg |R|] + 1 calls to (s,t)-mincut on weighted
graphs of O(n) vertices and O(m) edges, and takes O(m) deterministic time outside of
the mincut calls. If the original graph G is unweighted, then the inputs to the mincut
calls are also unweighted.

Our main idea is to first compute, for each terminal, an “upper boundary” to the location
of the mincut separating that terminal from the rest (see Figure . More precisely, for
each terminal v € R, we want to compute a set U, of vertices that contains S, as defined in
Definition . If we can do so, then it suffices to compute an (v, ¢)-mincut on the graph G
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with V' \ U, contracted to a single vertex t, which will return 95, or some other (v, R\ v)-
mincut. To make this mincut computation fast, we would like U, to be small, ideally not
much larger than S,. We are not able to prove such a strong local guarantee, but we can
ensure that the sets U, are disjoint among all v € R. In other words, some terminal v € R
might have small S, and linear-sized U,, but this cannot happen for too many terminals,
since ) . |Uy| < n must hold.

Our procedure to compute the sets U, is as follows. We first compute [lg|R|] many
bipartitions of R such that any two terminals are separated in at least one bipartition. For
each bipartition (A, B) of R, we compute a (A, B)-mincut, and then for each terminal v € R,
we set U, as the common intersection of the sides containing v of the [lg | R|] many computed
mincuts. We show by a simple submodularity argument that the side containing v of each of
the [lg |R|] mincuts must contain S, (if we assume S, to be minimal in a sense), and thus,
their common intersection U, also contains S,.

For the rest of this section, we formalize the above intuition and prove Lemma [2.2.2

Proof of the isolating cuts lemma (Lemma[2.2.2)). Order the vertices in R arbitrarily
from 1 to |R|, and let the label of each v € R be its position in the ordering, a number from 1
to | R| that is denoted by a unique binary string of length [lg |R|]. Let us repeat the following
procedure for each i = 1,2,..., [lg|R|]|. For each vertex v, color it red if the i’th bit of its
label is 0, and blue if the ’th bit of its label is 1. Then, compute a min-cut C; C F in G
between the red vertices and the blue vertices (for iteration 7).

First, we show that G'\ |J, C; partitions the set of vertices into connected components
each of which contain at most one vertex of R. Let U, be the connected component in

G\ U, C; containing v € R. Then:

U, R ={v} for all v € R.

Proof. By definition, v € U, N R. Suppose for contradiction that U, N R contains another
vertex u # v. Since the binary strings assigned to u and v are distinct, they differ in their
J'th bit for some j. Then, the cut C; must separate u and v, i.e., removing the edges in C;
leaves u and v in separate components, which is a contradiction. O

Now, for each vertex v € R, let A, be the minimum value of w(dS) over all S C V
satisfying SN R = {v}, and let S} C V be an inclusion-wise minimal set satisfying S} N R =
{v} and w(9S}) = A,. Then, we claim that the cut S} does not cross the cut U,, i.e.:

U, 2 5; for all v € R.

Proof. Fix a vertex v € V and an iteration 7. Let the side of the cut C; containing v be
T: C V; we claim that S¥ C T!. Suppose for contradiction that S} \ T # (). Note that
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(S*NTH N R = {v}, which implies that:
w(9(S*NTH) > N, = w(dST).
Indeed, by our choice of S} to be inclusion-wise minimal, we can claim the strict inequality:
w(0(SFNTY) > N\, = w(dS).
But, by submodularity of cuts, we have:
w(O(SEUTH) +w(0(S:NTH) <w(dSF) +w(0Tr).

Therefore, we get:

w(A(SFUTY) < w(OTh).

But (S UTH)NR=T'NR since (S:\T))N R = (. In particular, the cut 9(S; UT!) also
separates red vertices from blue vertices in the ith iteration. This contradicts the choice of
OT' = C; as the min-cut separating red vertices from blue vertices in the ith iteration.
Therefore, over all iterations 4, none of the edges in the induced subgraph GI[S}] are
present in C;. Note that G[S}] is a connected subgraph; therefore, it is a subgraph of the
connected component U, of G\ |J; C; containing v. O

It remains to compute the desired set S, given the property that U, O S,. Starting from
G, contract V'\ U, into a single vertex t; we want to compute the min v—¢ cut in the contracted
graph G,,, which corresponds to a set S, satisfying S,NR = {v} by Claim[2.2.3] Since 9, S; is
a valid v—t cut in this graph by Claim[2.2.4] we have w(9¢,S,) < w(9g,S;) = w(0cS;) = Ay,
as desired.

Note that each edge in E'is either in exactly one graph G,, or it is adjacent to ¢ in exactly
two graphs G,. Therefore, the total number of edges over all graphs G, is at most 2m. We
can compute the v—t min-cuts on all GG, in “parallel” through a single max-flow call on the
disjoint union of all G,. Note that if the original graph G is unweighted, then this max-flow
instance is also unweighted. Finally, recovering the sets S, and the values w(05,) take time
linear in the number of edges of G,, which is O(m) time over all v € R.

This completes the proof of Lemma [2.2.2] O

2.3 Conclusion

In just one year since its discovery, the minimum isolating cuts lemma has seen many appli-
cations in graph cut problems from Gomory-Hu tree (Chapter [4)) to vertex connectivity [73],
as well as more abstract problems like bisubmodular function minimization |23 84]. We
anticipate many more future applications to come, and we expect the technique to reshape
how we approach locality in graph algorithms in the same way expander decomposition did
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Chapter 3

Steiner Mincut

This chapter focuses on the (global) Steiner mincut problem: given an undirected graph and a
subset T of vertices (called the terminals), the (global) Steiner mincut is the smallest-weight
set of edges whose removal disconnects at least two vertices in T. The Steiner mincut is the
natural generalization of both the global mincut and (s, ¢)-mincut problems. Nevertheless,
prior to the results of this chapter, no nontrivial algorithm for Steiner cut was known in
general.

In this chapter, we show that the isolating cuts lemma from Chapter 2] leads to a simple
and elegant algorithm for Steiner mincut that uses polylogarithmic many calls to (s,t)-
mincut. For weighted graphs and unweighted graphs of large enough Steiner connectivity,
this is the fastest algorithm known. More importantly, since Steiner mincut also general-
izes (s,t)-mincut, we conclude that both problems have the same time complexity up to
polylogarithmic factors. We then derandomize our Steiner mincut algorithm using standard
derandomization tools along with expander decompositions. At the time of publication, our
algorithm was the fastest for even the special case of deterministic global mincut.

It is worth emphasizing again the simplicity of the randomized Steiner mincut algorithm.
The only step in addition to the isolating cuts algorithm of Chapter [2] is an initial random
sampling process. Namely, we sample terminals at varying sampling probabilities, and for
each sample, we compute the minimum isolating cuts on the sampled terminals. A simple
argument shows that with high probability, at least one of the isolating cuts computed over
all samples is the minimum Steiner mincut.

It is rather remarkable that such a simple yet state-of-the-art Steiner mincut algorithm
remained undiscovered for so long. Once again, we attribute our success to our locality
perspective: as mentioned in the beginning of Chapter[2], viewing the Steiner mincut problem
from a locality perspective is what led us to the minimum isolating cuts problem in the first
place!
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3.1 Background

For unweighted graphs, the fastest algorithm for Steiner mincut, due to Bhalgat et al. [16],
runs in O(m + nc?) time where ¢ is the size of the Steiner mincut. For weighted graphs,
nothing nontrivial was known before our result.

The Steiner mincut problem is an important subroutine for fast Gomory-Hu tree algo-
rithms. For example, a Gomory-Hu tree can be constructed using n — 1 calls to Steiner
mincut instead of (s,¢) max-flow. Bhalgat et al. [I6] follow this approach to compute a
Gomory-Hu tree, except they show that computation can be reused between the n — 1 calls
of their specific Steiner mincut algorithm, speeding up their Gomory-Hu tree algorithm to

O(mn) time in total. We remark that their speed-up methods do not apply to our Steiner
mincut algorithm, so we do not obtain a faster Gomory-Hu tree algorithm as a corollary.

3.2 Randomized Steiner Mincut

In this brief section, we show that the isolating cuts lemma (Lemma [2.2.2)), along with a
simple random sampling procedure, implies a randomized Steiner mincut algorithm that
makes polylog(n) many max-flow calls.

Theorem 3.2.1: Randomized Steiner mincut

There is a randomized, Monte Carlo algorithm for Steiner mincut for weighted undi-
rected graphs that makes O(log® n) calls to (s,t) max-flow on a weighted, undirected
graph with O(n) vertices and O(m) edges. If the original graph G is unweighted, then
the inputs to the max-flow calls are also unweighted.

Proof. The algorithm essentially calls Lemma O(log® n) times; in each iteration, R C T
is a random set of vertices sampled at a particular scale.

For each positive integer i < lg|T'|, repeat the following procedure O(logn) times: let
R C V be a random sample of 2 vertices, and call Lemma on the set R to obtain a
cut S, for each v € R. Return the cut S with the minimum value of w(9S,) over all v and
over all the iterations.

We claim that w.h.p., the returned cut S is a Steiner mincut. Let S* C V be the side
of the Steiner mincut containing the smaller number of terminals. Observe that if, in any
iteration, the sampled set R satisfies |R N S*| = 1, then Lemma will find the Steiner
mincut. Consider the integer ¢ = |1g(n/|S*|)]. Then, for each iteration where R is a random
sample of size 2¢, we sample exactly one vertex in S* with probability ©(1). Since we sample
at this scale O(logn) times, this occurs at least once w.h.p. ]
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3.3 Deterministic Steiner Mincut

In this section, we present our deterministic Steiner mincut algorithm.

Theorem 3.3.1: Deterministic Steiner mincut

Fix any constant € > 0. There is a deterministic algorithm for global Steiner mincut
that makes (Ign)?1/<) calls to (s,t) max-flow on a weighted undirected graph with
O(n) vertices and O(m) edges, and runs in O(m!'*€) time outside these max-flow calls.
If the original graph G is unweighted, then the inputs to the max-flow calls are also
unweighted.

Throughout the algorithm, we maintain a set U C T of vertices that starts out as U =T
and shrinks over time. (Think of this set as the set R over which we call the isolating cuts
lemma.) We distinguish between the cases when U is k-unbalanced or k-balanced for some
k = polylog(n), as defined below.

Definition 3.3.2: k-unbalanced, k-balanced

For any positive integer k, a subset U C T is k-unbalanced if there exists a side S C V
of some Steiner mincut satisfying 1 < [SNU| < k. More specifically, we say that U is
k-unbalanced with witness S. The subset U C T is k-balanced if there exists a Steiner
mincut whose two sides Sy, Sy satisfy |S; N U| > k for both i = 1,2. More specifically,
we say that U is k-balanced with witness (Si, Ss).

We will only use this definition for subsets U C T that span both sides of some Steiner
mincut, i.e., SNU # 0 and (V' \ S)NU # @ for some Steiner mincut S. By definition, such
a subset U C T is either k-unbalanced or k-balanced (or possibly both, if there are multiple
Steiner mincuts in the graph). If U is k-unbalanced with witness S for some k = polylog(n),
then the algorithm computes a family F of subsets of U of size k°Mpolylog(n) = polylog(n)
such that some subset R € F satisfies |[R N'S| = 1. The algorithm then executes the
isolating cuts lemma (Lemma on each subset in F, guaranteeing that the target set
R is processed and the Steiner mincut is found. Otherwise, U must be k-balanced with
some witness (S7,953). In this case, the algorithm computes a subset U’ C U such that
|U’| < |U|/2 and both S;NU" # () and So N U’ # 0. Of course, the algorithm does not
know which case actually occurs, so it executes both branches. But the second branch can
only happen O(logn) times before |U| < k, at which point we can simply run (s,¢) min-cut
between all vertex pairs in U.

The algorithm is presented in Algorithm [I}

3.3.1 Unbalanced Case

In this section, we solve the case when U is k-unbalanced (line for some fixed k =
polylog(n).
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Algorithm 1 Deterministic Steiner mincut on (G = (V, E))
U<+ T
k < C'log® n for a sufficiently large constant C' = O(1/€*)
while |[U| >k do
Run Lemma [3.3.3 on U > Handles case when U is k-unbalanced (see
Definition
Compute U’ from U according to Lemma [3.3.7]> Handles case when U is k-balanced
Update U < U’ > |U| shrinks by at least factor 2

for each pair of distinct s,¢t € U do
Compute min (s,t) cut in G

return smallest cut seen in lines M4l and

Lemma 3.3.3: Unbalanced case

Consider a graph G = (V, E), a parameter k > 1, and a k-unbalanced set U C T'. Then,
we can compute the Steiner mincut in k°Mpolylog(n) (s,t) max-flow computations
plus O(m) deterministic time.

Our goal is to de-randomize the simple random process of sampling each vertex indepen-
dently with probability 1/k. We compute a deterministic family of subsets R C U such that
for any subset S satisfying |S N U| < k (in particular, for the Steiner mincut witnessing the
fact that U is k-unbalanced), there exists a subset R in the family with |[RN.S| = 1. We call
such a subset R an isolator.

Lemma 3.3.4: Deterministic isolator

For every n and k < n, there is a deterministic algorithm that constructs a family F
of subsets of [n] such that, for every non-empty subset S C [n] of size at most k, there
exists a set T € F with |[SNT| = 1. The family F has size k°®) log n, every set in the
family has at least two elements, and the algorithm takes k°Mnlogn time.

. J

Before we prove Lemma [3.3.4] we first show why it implies an algorithm for the unbal-
anced case as promised by Lemma [3.3.3
Proof of Lemma[3.3.3. Let S be the Steiner mincut witnessing the fact that U is
k-unbalanced. Apply Lemma with parameters n = |U| and k. Map the elements
of [n] onto U, obtaining a family F of subsets of U such that for any set S’ C U with
|S"| < k, there exists a set R € F with |R| > 2 and |[RN 5’| = 1. In particular, for the
set S" = SNU, there exists R € F with 1 = |[RN S| = |RN(SNU)| =|RNS|. Invoke
Lemma on the set R to obtain, for each v € R, a set S, satisfying S, N R = {v} that
minimizes w(9S,), along with the value w(95S,). Finally, output the set S, with minimum
value of w(9S,). To show that S, is a Steiner mincut of graph G, it suffices to verify that
Sy is a valid cut (that is, § € S, C V'), and that w(9S,) < w(99).
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Since |R| > 2, the set S, satisfies ) C S, C R, so it is a cut of the graph G. Since
|IRN S| = 1, for the vertex u € U with RN S = {u}, the set S satisfies the constraints
for S,. In particular, w(9S,) < w(dS). We output the set S, minimizing w(dS,), so
w(0S,) < w(9S,) < w(dS), as promised. O

The rest of this section focuses on proving Lemma We first prove an easier variant,
where we do not insist that every set in the family has at least two elements.

Lemma 3.3.5: Deterministic isolator, singletons allowed

For every n and k, there is a deterministic algorithm that constructs a family F of

subsets of [n] such that, for each subset S C [n] of size at most k, there exists a set

T € F with [SNT| = 1. The family F has size k< logn and the algorithm takes

k°Wnlogn time.

To prove Lemma we use the following de-randomization building block due to [7].
The theorem below is from [31], who state it in terms of (n, k, k?)-splitters (which we will
not define here for simplicity).

Theorem 3.3.6: Deterministic splitters (Theorem 5.16 from [31])

For any n,k > 1, one can construct a family of functions from [n] to [k?] such that for
every set S C [n] of size k, there exists a function f in the family whose values f(7)
are distinct over all i € S. The family has size k() logn and the algorithm takes time
k°Wnlogn.

Proof of Lemma[3.3.5. Apply Theoremto n and k, and for each function f : [n] — [£k?]
in the constructed family, add the sets f~!(j) for all j € [k*] to our family F of subsets of
[n]. Fix any set S C [n] of size k. For the function f guaranteed by Theorem for this
set S, we have |f~1(f(i)) NS| =1 for any i € S. Therefore, setting T' = f(i) for any i € S
suffices.

This only handles subsets S C [n] of size exactly k, but we can repeat the above con-

struction for each positive integer k' < k. The total size and running time go up by a factor
of k, which is absorbed by the k2™ factors. O

Finally, to prove Lemma [3.3.4] we add the condition that F cannot contain sets of size
at most 1. Here, we will impose the additional constraint that & < n.
Proof of Lemma|3.5.4). The only difference in the output is that F must contain no sets of
size at most 1. Apply Lemma to n and k to obtain a family Fy. Initialize a set F as
Fo minus all subsets of size at most 1. For each singleton set {z} € Fy, choose k arbitrary
elements in [n] \ z, and for each chosen element y, add the set {x,y} to F. The total size
of F increases by at most a factor k. Now consider a subset S C [n] of size at most k, and
let T be a set in Fy with [SNT| =1, as promised by Lemma [3.3.5] If [T'| > 1, then T € F
as well. Otherwise, if 7' = {z}, then since |S \ | < k and we chose k elements y € [n] \ z,
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there exists some chosen y ¢ S for which {z,y} was added to F. This set {x,y} satisfies
S0 {ry) = 1. .

3.3.2 Balanced Case: Sparsifying U

If U is k-balanced, then we compute a subset U’ C U of size at most |U|/2 using expander
decompositions, while preserving the condition that U’ spans both sides of some Steiner
mincut. This section is dedicated to proving the following lemma:

Lemma 3.3.7: Deterministic sparsification of U

Fix any constant € > 0. Then, there is a constant C' = O(1/¢?) such that the following
holds. Consider a graph G = (V, E), a parameter ¢ < 1/(C'log®n), and a set U C V
of vertices that is (14 1/¢)3-balanced with witness (S}, S2). Then, we can compute in
deterministic O(m!*) time a set U’ C U with |U’| < |U]|/2 such that S; N U’ # () for
both i =1, 2.

Deterministic expander decomposition. Our main tool will be deterministic expander
decompositions with custom demands, a generalization of standard expander decompositions.
We first introduce some new notation. Let G = (V| F) be a weighted, undirected graph. For
disjoint vertex subsets Vi,...,V; C V| define E(V3,...,V,) as the set of edges (u,v) € E
with v € V; and v € V; for some ¢ # j. Recall that w(F) is the sum of weights of edges
in F;ie, w(E(V,...,V,)) is the sum of weights of edges with endpoints in different vertex
sets in V1, Vs, ..., V4. In particular, for a cut (A, B), we denote the edges in the cut both by
E(A, B) as well as the previously introduced notation 0A (or dB), and the weight of the cut
is correspondingly denoted w(E(A, B)) as well as w(0A) (or w(dB)). For a vector d € R
of entries on the vertices, define d(v) as the entry of v in d, and for a subset U C V', define
d(U) := >, pd(v).

We now introduce the concept of an expander “weighted” by custom demands on the

vertices.

Definition 3.3.8: (¢, d)-expander

Consider a weighted, undirected graph G = (V, F) with edge weights w and a vector
de R‘z/o of non-negative “demands” on the vertices. The graph G is a (¢, d)-expander
if for all subsets S C V/,
w(9S)
: > .
min{d(S),d(V \ 5)}

. J

Intuitively, to capture the intersection of a set with U, we will place demand A at each
vertex v € U, where A is the weight of the Steiner mincut, and demand 0 at the remaining
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vertices. We now state the deterministic algorithm of [27] that computes our desired expander
decomposition.

Theorem 3.3.9: Deterministic (¢, d)-expander decomposition

Fix any constant € > 0 and any parameter ¢ > 0. Given a weighted, undirected graph
G = (V, E) with edge weights w and a non-negative demand vector d € RY, on the
vertices, there is a deterministic algorithm running in O(m!*¢) time that partitions V
into subsets V7, ..., V, such that
1. For each i € [¢], define the demands d; € RY as d;(v) = d(v) +w(E({v}, V\V}))
for all v € V;. Then, the graph G[V}] is a (¢, d;)-expander.

2. The total weight w(E(Vi,...,V;)) of inter-cluster edges is Bod(V) where B =
(Ig )00/,

Sparsification Algorithm. Let \ € [\, 3\] be a 3-approximation to the Steiner mincut
value A, which can be computed in deterministic O(m) time using the (24 §)-approximation
algorithm of Matula (for any ¢ > 0) [80]. Set ¢ := 1/(Clog® n) for a sufficiently large con-
stant C' > 0, and let € > 0 be the constant fixed by Theorem [3.3.1] We apply Theorem [3.3.9]
to G with parameters €, ¢ and the demand vector d € RY, satisfying d(v) = AforallveU
and d(v) = 0 for all v € V' \ U. Observe that d(V) = |U|- X < [U]-3X\. Let V4,...,V, CV
be the output, and for each i € [¢], define U; :=V; N U.

We now describe the procedure to select the subset U’ C U. We say that a cluster V;
is trivial if U; = 0, small if 1 < |U;] < 1/¢?, and large if |U;| > 1/¢*. The algorithm for
selecting the set U’ is simple:

— for each trivial cluster, do nothing;

— for each small cluster V;, add an arbitrary vertex of U; to U’;

— for each large cluster V}, add 1+ 1/¢ arbitrary vertices of U; to U’.

Size bound. First, we prove the desired size bound of the sparsified set U’, which is one
part of Lemma [3.3.7]

Claim 3.3.10: Number of clusters

There are at most Bo|U| many clusters; that is, £ < B¢|U| where B = (g n)0(1/€4).

Proof. Since A is the Steiner mincut value of graph G, each cluster V; has w(0V;) > A, so
the total weight of inter-cluster edges is at least £A/2. By the guarantee of Theorem [3.3.9]
the total weight of inter-cluster edges is at most

Bod(V) = Bo|U|X < Bo|U|),
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where B = (Ign)°1/<) Putting these together gives ¢ < B@|U| as desired. O

Corollary 3.3.11: Size bound

There exists a constant C' = O(1/€*) such that if ¢ < 1/(Clog®n), then the set U’
constructed by the sparsification algorithm satisfies |U’| < |U|/2.

Proof. There are at most B¢|U| small clusters by Claim [3.3.10, Also, there are at most
®?|U| large clusters since each large cluster has at least ¢? vertices in U. This gives

0" < Bo|U| + ¢*|U| - (1 + 1/¢)
< O(Bo|U) < ¢|U] - 108; n

for an appropriate constant C' = O(B) = O(1/€*). Since ¢ < 1/(C'log® n), we have
U] < ¢|U] - log n < |U|/2.
]

Hitting both sides of the Steiner mincut. Now, we prove the “hitting” property of the
sparsified set U’ in Lemma namely the guarantee that S; N U’ # () for both 7 = 1, 2.

The claim below says that the Steiner mincut (A, B) cannot cut too “deeply” into the
sets U;. In particular, if a set U; is large (say, |U;| > 1/¢), then the Steiner mincut cannot
cut U; evenly in the sense that |U;NA| = |U; N B; instead, we either have |U;NA| < |U;N B|
or |[U;NA|>|U;N Bj.

Claim 3.3.12

For any cut (A, B) of G, we have

w(E(A, B))

€[4

where U; :==V; N U for i € [{].

Proof. Since G[Vi] is a (¢, d;)-expander, and since d;(S) > d(S) = |[UNS|-A>|UNS|- A
for all subsets S C V;, we have

w(B(V; N A,V N B))
min{|U N (V; A A)[- M U A (V; " B)[- A}

w(E(V;N A, V;NB))
~ min{d;(U; N A),d;(U; N B)

}_cb
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This means that
min{|U; N A| - A, |U; N B| - A}

=min{|[UN(V;NA)|-\|UN(V;NB)|- A}
< w(E(U; NA,U;N B))
- ¢
Since E(V; N A,V; N B) is contained in F(A, B) and is disjoint over all i, we have

> w(BE(V;N A, V;NB)) < w(E(A, B)).

1€[{]
Putting things together,
> min{|U; N A, |U; N Bl}

1€[{]
I —w(EWV;NAV,NB))
<3 % 5
_ w(B(A,B))
< o .

O

We say that a cut C' cuts a cluster V; if both C'NV; and V; \ C are non-empty. The next
claim states that the Steiner mincut can only cut a few clusters V;, i.e., only a few clusters V;
overlap both sides of the Steiner mincut. This implies that for the sets U; C V; in particular,

all but a few of them satisfy U;N A =0 or U; N B = .

Let C be one side of a Steiner mincut (i.e., w(9C) = A). Then, C cuts at most (1+1/¢)

clusters V;.

Proof. Suppose for contradiction that C' cuts more than (1 + 1/¢) clusters. Fix a cluster V;
that is cut, and let A; and B; be CNV; and V;\C (possibly swapped) so that w(FE(A4;, V\V;)) <
w(E(B;, V\V;)). The edges E(A;, B;) are contained in 0C, and across different clusters V;

that are cut, the edges E(A;, B;) are disjoint, so

> w(E(A;, B))) < w(9C) = .

Since C' cuts more than (1 + 1/¢) clusters, there exists a cluster V; with

w(@C) A
L+1/¢p 1+1/¢
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For all subsets S C V;, we have

di(S) = Y w(B({v}, V\ V) = w(E(S,V\ ;).

veS
Since G[V] is a (¢, d;)-expander,
w(E(A;, By))
> ¢ - min{w(E(A;, V\ Vi), w(E(B.V \ Vi))}
= ¢ w(E(A;, V\V))).

Consider the cut 0A;, which satisfies
w(0A;) = w(E(A;, B)) + w(E(A;, V\ V)

< w(E(A, B) + %w(E(A@-, B,))

_ <1 i é) w(E(A; By)) < A

contradicting the fact that C' is the Steiner mincut. n

Finally, we prove the “hitting” property of the sparsified set U’. This, along with Corol-
lary (3.3.11] finishes the proof of Lemma

Lemma 3.3.14: Hitting property

Suppose that U is (1 + 1/¢)3-balanced with witness (51, S2). Then, for the set U’
constructed by the sparsification algorithm, we have S; N U’ # () for both 7 = 1, 2.

Proof. For each cluster V;, by Claim [3.3.12]

w(B(4,B) _1
PA 9
In other words, either |S; NU;| < 1/¢ or | Sy NU;| < 1/¢. Call a cluster V;:
1. white if S;NU; =0 (ie., U; € Ss).
2. light gray if 0 < |S; N U;| < |S2 NU;| < |U;], which implies that 0 < |[S; N U;| < 1/¢.
3. dark gray if 0 < [Sy NU;| < |S1 NU;| < |U;|, which implies that 0 < [So NU;| < 1/¢.
4. black if SoNU; =0 (i.e., U; C Sy).
Every cluster must be one of the four colors, and by Claim there are at most (1+1/¢)

(light or dark) gray clusters since U; NSy, U; N Sy # 0 implies that S; cuts cluster V;. Note
that since we are only considering clusters V; such that U; # (), it must be that for a white

min{|U; N A, |U; N B[} <
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cluster, we have | Sy NU;| # 0, and similarly, for a black cluster, we have |S;NU;| # (). There
are now a few cases:

1. There are no large clusters. In this case, if there is at least one white and one black
small cluster, then the vertices from these clusters added to U’ are in S, and Si,
respectively. Otherwise, assume w.l.o.g. that there are no black clusters. Since there
are at most (14 1/¢) gray clusters in total, |S; NU| < (14 1/¢) - 1/¢?, contradicting
our assumption that min{|S; NU|,|Se N U} > (1 + 1/¢)3.

2. There are large clusters, but all of them are white or light gray. Let V; be a large
white or light gray cluster. Since we select 1 + 1/¢ vertices of U;, and |S; N U;| =
min{|S1 NU;l,|SeNU;|} < 1/¢, we must select at least one vertex not in Sy. Therefore,
Sy MU’ # (. If there is at least one black cluster, then the selected vertex in there is
in U’, so S;NU" # () too, and we are done.

So, assume that there is no black cluster. Since all large clusters are light gray (or
white), |S1 NU;| < 1/¢ for all large clusters V;. Moreover, by definition of small
clusters, [S1 NU;| < |U;| < 1/¢? for all small clusters V;. Since there are at most

(14 1/¢) gray clusters by Claim [3.3.13

7:V; small i:V; large

<(1+1)-i+(1+1).l
- o) ¢ ¢) ¢

1\ 1 1\*
”(”a)f(”a) ’

3. There are large clusters, but all of them are black or dark gray. This is symmetric to
case (2) above with S; replaced with Ss.

a contradiction.

4. There is at least one black or dark gray large cluster V;, and at least one white or
light gray large cluster V;. In this case, since we select 1 4 1/¢ vertices of U; and
|So N U;| = min{|S; NU;|, |S2 NU|} < 1/¢, we must select at least one vertex in Sj.
Similarly, we must select at least one vertex in U; that is in Ss.

O

3.4 Conclusion

In this chapter, we established an equivalence between the Steiner mincut and (s, ¢)-mincut
problems, up to polylogarithmic factors for randomized algorithms and n°®) factors for de-
terministic. One immediate question is whether the deterministic reduction can improved to
a polylogarithmic overhead. With the current approach, this essentially reduces to whether
deterministic expander decompositions can be computed with polylog(n) guarantees every-
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where. As previously discussed in Section such an improvement would require sig-
nificantly new ideas. Alternatively, it may be possible to use more powerful tools from
de-randomization to bypass expander decompositions altogether.

On the applications side, the Steiner mincut was historically studied in the context of
computing a Gomory-Hu tree [16]. It had not seen much action elsewhere due to the pro-
hibitive running time of past algorithms for the problem. Now that Steiner mincut can
be solved much more efficiently, we expect more algorithms to use Steiner mincut as an
important primitive.
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Chapter 4

Gomory-Hu Tree

In this section, we discuss our algorithms for computing a Gomory-Hu tree [71], a classic
data structure that encodes all pairwise (s,t)-mincuts in the form of a single tree. That
is, an algorithm computing the Gomory-Hu tree can also answer all-pairs (s, ¢)-mincuts,
which is itself a fundamental problem on graphs. We approach the Gomory-Hu tree problem
from a locality perspective, combining the isolating cuts lemma (Lemma and random
sampling in a similar way to the Steiner mincut algorithm of Section [3.2]

Our Gomory-Hu tree algorithms resemble Gomory and Hu’s original algorithm, which
iteratively computes (s,t)-mincuts and applies recursion on each side. However, instead of
computing a single (s, t)-mincut at each step, we compute multiple (s, ¢)-mincuts for a fixed
source s, ensuring that we make enough progress at each recursive step. This is where the
minimum isolating cuts algorithm becomes useful: it computes an isolating cut around each
terminal, giving us many candidate (s,t)-mincuts. However, the difficulty is in verifying
which of these cuts are truly (s,¢)-mincuts for some ¢ and which are not. In fact, we do
not know a verification procedure beyond trivially computing the (s, ¢)-mincut for each t.
Nevertheless, while we do not obtain a faster (exact) Gomory-Hu tree at the end, we are
able to reduce the problem down to this verification step, which we call single-source mincut
verification. This is a significant step towards obtaining faster Gomory-Hu tree algorithms,
since future endeavors at the Gomory-Hu tree problem can focus instead on this simpler,
seemingly more tractable single-source mincut verification.

If we are happy with an approrimate Gomory-Hu tree, however, the verification step
can be bypassed entirely, leading to an algorithm in roughly max-flow time. To obtain
this algorithm, we define a core subroutine called the Cut Threshold problem: we are given
a source vertex and a real number A called the cut threshold, and we want to output all
vertices whose mincut from the source is at least A\. We solve this problem through minimum
isolating cuts in a similar manner as above, and then apply the Cut Threshold subroutine to
obtain the approximate Gomory-Hu tree. Since our result, the Cut Threshold problem itself
has already seen another application to the edge-augmentation problem [19], suggesting that
it is a fundamental problem in its own right.
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4.1 Background

Gomory-Hu trees originated from a classic result of Gomory and Hu [44], who showed that
by using just n — 1 max-flows, they could construct a tree T" on the vertices of an undirected
graph G such that for every pair of vertices s and ¢, the (s,t) edge connectivity in 7" was
equal to that in G. In other words, the (;‘) pairs of vertices had at most n — 1 different edge
connectivities and they could be obtained using just n — 1 max-flow calls.

However, despite rapid advancements in cut and flow algorithms since then, the best
algorithm for constructing a GH-tree remains the one given by Gomory and Hu almost six
decades after their work. There have been alternatives suggested along the way, although
none of them unconditionally improves on the original construction. Bhalgat et al. [I7] (see
also [50]) obtained an O(mn) algorithm for this problem, but only for unweighted graphs,
and Abboud et al. [2] improved this bound for sparse unweighted graphs to O(m3/ 2p1/%).

Due to the intractability of the general Gomory-Hu tree problem, there has been recent
action on obtaining approzimate results. In a beautiful paper, Abboud et al. [3] showed that
the problem of finding all pairs edge connectivities (that a GH tree obtains) can be reduced
to polylog(n) instances of the single source mincut problem: given a fixed source vertex s,
find the (s,t)-mincut of s with every other vertex t. They solve the single source mincut
problem in O(n?) time by calling n — 1 approximate max-flows on an O(n)-sized sparsifier
of the graph, leading to a total running time of O(n2) However, they do not recover an
approximate Gomory-Hu tree; indeed, prior to our work, no nontrivial result on approximate
Gomory-Hu trees were known.

4.2 Our Results

We first study the traditional Gomory-Hu tree problem, defined below.

Definition 4.2.1: Gomory-Hu tree

Given an undirected graph G = (V| F), a Gomory-Hu tree is a weighted tree 7" on V
such that
e For all s,t € V, consider the minimum-weight edge (u, v) on the unique s—t path
in T. Let U’ be the vertices of the connected component of T'— (u, v) containing
s. Then, the set U’ C V is an (s,t)-mincut, and its value is the weight of the
(u,v) edge in T

Our first result is a reduction from the Gomory-Hu tree problem to essentially verifying
a collection of (s,t)-mincuts sharing a common vertex s. We call this problem single-source
mincut verification.
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Definition 4.2.2: Single-source mincut verification

The input to single-source mincut verification is a graph G = (V, E), a source vertex
s € V, and a value A\, for each v € V'\ s such that A\, > mincut(s,v). The task is to
determine, for each vertex v € V'\ s, whether or not A\, = mincut(s,v).

Theorem 4.2.3: Gomory-Hu tree reduces to single-source mincut verification

There is a randomized algorithm that outputs a Gomory-Hu tree of a weighted, undi-
rected graph w.h.p. It makes calls to single-source mincut verification on graphs with
a total of O(n) vertices and O(m) edges, and runs for max-flow time outside of these
calls.

Clearly, single-source mincut verification is no harder than computing a Gomory-Hu tree,
so the theorem above shows that the two problems are equivalent. Therefore, future efforts
at obtaining a faster Gomory-Hu tree algorithm may be directed instead at the single-source
mincut verification problem, which seems simpler and more tractable.

Unfortunately, we do not know how to solve single-source mincut verification faster than
the O(mn) time algorithm of Bhalgat et al. [I6]. However, if we resort to approximations,
then we can do better, even on weighted graphs. We define the approximate version of
Gomory-Hu tree below.

Definition 4.2.4: Approximate Gomory-Hu tree

Given an undirected graph G = (V, E), a (1 + €)-approximate Gomory-Hu tree is a
weighted tree 7" on V' such that
e For all s,t € V, consider the minimum-weight edge (u, v) on the unique s—t path
in T. Let U’ be the vertices of the connected component of 7' — (u, v) containing
s. Then, the set U' C V is a (1 + ¢€)-approximate (s,t)-mincut, and its value is
the weight of the (u,v) edge in T

Theorem 4.2.5: Approximate Gomory-Hu tree in max-flow time, weighted

Given a weighted, undirected graph, there is a randomized algorithm that w.h.p.,
outputs a (1 + €)-approximate Gomory-Hu tree and runs in O(m) time plus calls to
exact max-flow on instances with a total of O(ne™!) vertices and O(ne ') edges. Using
the O(my/n) time max-flow algorithm of Lee and Sidford [65], the algorithm runs in
O(m + n3/?e¢=19) time.

For unweighted graphs, we obtain the following result, which gives a better running time
for sparse graphs, assuming state-of-the-art max-flow algorithms.
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Theorem 4.2.6: Approximate Gomory-Hu tree in max-flow time, unweighted

Let G be an unweighted, undirected graph. There is a randomized algorithm that
w.h.p., outputs a (1 + €)-approximate Gomory-Hu tree and runs in O(m) time plus
calls to exact max-flow on unweighted instances with a total of O(ne~!) vertices and
O(me™") edges. Using the m*/3t°()_time max-flow algorithm for unweighted graphs

of Liu and Sidford [75], the algorithm runs in m*3+°Me=4/3 time.

We emphasize that we are reducing an approzimate problem to calls of ezact max-flow,
which is an important distinction in the proof. It is an open problem whether we can reduce
to approximate max-flow instead, which is solvable in near-linear time [96].

The approximate Gomory-Hu tree algorithms solve the following new problem as a sub-
routine. It looks basic enough that it may have future applications elsewhere, so we include
its definition and corresponding result in this introductory section.

Definition 4.2.7: Cut threshold

Given an undirected graph, a source vertex s € V, and a value A > 0 called the
cut threshold, the cut threshold problem asks to compute all vertices v € V '\ s with
mincut(s,v) < A.

Theorem 4.2.8: Cut Threshold in max-flow time

There is an algorithm solving the cut threshold problem that runs in O(m) time plus
polylog(n) calls to max-flow instances on O(n)-vertex, O(m)-edge graphs.

4.3 Owur Techniques

To sketch our main ideas, let us first think of the CT problem (Theorem [£.2.8). Note that
this theorem is already sufficient to obtain the improved the running times for the SSMC
and APMC problems, although obtaining a (1 + €)-approximate GH tree needs additional
ideas. To solve the CT problem, our main tool is the isolating cuts lemma from Chapter 2]
We actually need a slightly stronger version of it in this chapter that guarantees minimal
(v, R\ v)-mincuts, along with a disjointness property. The proof is almost unchanged from
the original isolating cuts lemma (Lemma , so we skip the proof.

Note that this time, we require calls to (s,t) max-flow instead of (s, t¢)-mincut. This is
because a minimal (s, t)-mincut can be recovered from an (s,¢) max-flow in linear time. The
change makes no difference in practice, since the state-of-the-art (s,¢) max-flow and mincut
running times are the same.

We first describe our strategy for the reduction from GH tree to SSMC verification. For
now, fix an arbitrary source vertex s. Our goal is to compute a collection of disjoint sets
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S; C V such that each S; is an (s,v)-mincut for some v with |S;| < n/2, and a total size
guarantee of > |S;[ = Q(n/logn). If we can do so, then we can split the graph along the
mincuts similarly to the standard recursive GH tree algorithm, obtaining recursive instances
of size at most n — Q(n/logn) each. This is enough to obtain a small recursion depth. (Of
course, we need to solve a Steiner version of GH tree in the recursive instances, but we leave
out that detail in this techniques section.)

Fix a GH tree T of the graph rooted at s, and let v be a vertex for which the subtree T, of
T rooted at v is an (s, v)-mincut with n, < n/2 vertices. Suppose we sample a set of vertices
from V' \ s at rate 1/n, and define this sample with s added to it as R. Then, we invoke
the isolating cuts lemma with this set R, obtaining disjoint sets 5, for each u € R. We
call SSMC verification to check whether each set S, is an (s, u)-mincut, and we separately
check whether |S,| < n/2, and if both checks pass, then we add S, to our collection of
subsets and mark all vertices in 5, as added. How many vertices do we end up marking?
For simplicity, let us first ignore the |S,| < n/2 check. Consider a vertex v and the subtree
T, of T rooted at v. With constant probability, exactly one vertex from 7;, is sampled in R,
and with probability €2(1/n,), this sampled vertex is v itself. In that happens, the isolating
cuts lemma would return the s — v mincut, namely the cut represented by the edge (u,v)
in the GH tree, and this cut S, would pass the SSMC verification check. This allows us
to mark the n, vertices in T,. So, roughly speaking, we are able to mark n, vertices with
probability 1/n, in this case, so on average, a vertex in 7;, is marked with at least constant
probability. Of course, we do not know the value of n,, but we separately try all sampling
levels in inverse powers of 2 and choose the sampling level with the most marked vertices.
We formalize and refine this argument to show that there is a sampling level for which we
can indeed mark Q(n/logn) vertices in expectation. Finally, we use an argument from [3]
that if s € V' is chosen uniformly at random, then the additional |S,| < n/2 check still allows
us to mark Q(n/logn) vertices in expectation.

The algorithm for the cut threshold (CT) problem is similar, except that instead of
performing the |S,| < n/2 and SSMC verification checks, we simply check whether w(9S,,) <
A. We then use the CT algorithm as a “sieve” to obtain an approximate SSMC algorithm for
a given source s, which is to compute a (1 + €)-approximate (s, v)-mincut for each v € V'\ s.
We start with mincut(s, v) for all vertices v € V'\ {s} tentatively set to the maximum possible
edge connectivity (call it Apay). Next, we run the CT algorithm with A = (1 — €)Apax. The
vertices v that are identified by this algorithm as having mincut(s,v) < A drop down to the
next level of the hierarchy, while the remaining vertices v’ are declared to have mincut(s, v') €
((1 — €)X, A]. In the next level of the hierarchy, we again invoke the CT algorithm, but now
with A equal to (1—e¢) factor of the previous iteration. In this manner, we iteratively continue
moving down the hierarchy, cutting the threshold A by a factor of (1 — €) in every step, until
the connectivity of all vertices has been determined.

Finally, we come to the problem of obtaining an approximate GH tree. The major
difficulty we face, compared to the exact GH tree case, is controlling the errors that propagate
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in the recursive algorithm. Gomory and Hu’s original algorithm uses the following strategy:
find an (s, t)-mincut for any pair of vertices s and ¢, and recurse on the two sides of the cut in
separate subproblems where the other side of the cut is contracted to a single vertex. They
used submodularity of cuts to show that contracting one side of an (s,t)-mincut does not
change the connectivity between vertices on the other side. Moreover, they gave a procedure
for combining the two GH trees returned by the recursive calls into a single GH tree at
the end of the recursion. Ideally, we would like to use the same algorithm but replace an
exact (s,t)-mincut with an approximate one. But now, the connectivities in the recursive
subproblems are (additively) distorted by the approximation error of the (s,t)-mincut. This
imposes two additional restrictions. (a) First, the values of the (s, t)-mincuts identified in
the recursive algorithm must now be monotone non-decreasing with depth of the recursion
so that the approximation error on a larger (s, t)-mincut doesn’t get propagated to a smaller
s" — t' mincut further down in the recursion. (b) Second, the depth of recursion must now
be polylog(n) so that one can control the buildup of approximation error in the recursion
by setting the error parameter in a single step to be e¢/polylog(n). Unfortunately, neither of
these conditions is met by Gomory and Hu’s algorithm. For instance, the recursion depth
can be n — 1 if each (s,t)-mincut is a degree cut. The order of (s,t)-mincut values in the
recursion is also arbitrary and depends on the choice of s and ¢ in each step (which itself is
arbitrary).

Let us first consider condition (a). Instead of finding the (s, t)-mincut for an arbitrary
pair of terminal vertices s and t, suppose we found the Steiner mincut on the terminals,
i.e., the cut of smallest value that splits the terminals. This would also suffice in terms
of the framework since a Steiner mincut is also an (s, ¢)-mincut for some pair s,¢. But, it
brings additional advantages: namely, we get the monotonicity in cut values with recursive
depth that we desire. At a high level, this is the idea that we implement: we use the CT
algorithm (with some technical modifications) where we set the threshold A to the value of
the Steiner mincut, and identify a partitioning of the terminals where each subset of the
partition represents a (1 + €) approximation to the Steiner mincut.

But, how do we achieve condition (b)? Fixing the vertex s in the invocation of the
SSMC algorithm, we can identify terminal vertices v that have mincut(s,v) € ((1 — €)A, Al
where A is the Steiner mincut. But, these approximate Steiner mincuts might be unbalanced
in terms of the number of vertices on the two sides of the cut. To understand the problem,
suppose there is a single Steiner mincut identified by the CT algorithm, and this cut is
the degree cut of s. Then, one subproblem contains all but one vertex in the next round
of recursion; consequently, the recursive depth can be high. We overcome this difficulty in
two steps. First, we ensure that the only “large” subproblem that we recurse on is the one
that contains s. This can be ensured by sampling O(logn) different vertices as s, which
boosts the probability that s is on the larger side of an unbalanced approximate Steiner
mincut. This ensures that in the recursion tree, we can only have a large recursive depth
along the path containing s. Next, we show that even though we are using an approximate
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method for detemining mincuts, the approximation error only distorts the connectivities in
the subproblems not containing s. This ensures that the approximation errors can build
up only along paths in the recursion tree that have depth O(logn). Combining these two
techniques, we obtain our overall algorithm for an approximate GH tree.

4.4 Additional Preliminaries

For our algorithm, it is more convenient to work with Gomory-Hu Steiner trees, which are
more amenable to our recursive structure.

Definition 4.4.1: Gomory-Hu Steiner tree

Given a graph G = (V, E) and a set of terminals U C V', the Gomory-Hu Steiner tree
is a weighted tree T" on the vertices U, together with a function f : V — U, such that
e For all s,t € U, consider the minimum-weight edge (u,v) on the unique s—t path
in T'. Let U’ be the vertices of the connected component of T'— (u, v) containing

s. Then, the set f~1(U’) C V is an (s,t)-mincut, and its value is wr(u,v).

Definition 4.4.2: Approximate Gomory-Hu Steiner tree

Given a graph G = (V, E) and a set of terminals U C V, the (1 + €)-approximate
Gomory-Hu Steiner tree is a weighted tree T" on the vertices U, together with a function
f:V — U, such that
e For all s,t € U, consider the minimum-weight edge (u, v) on the unique s—¢ path
in T. Let U’ be the vertices of the connected component of T'— (u, v) containing
s. Then, the set f~}(U’) C V is a (1 + ¢)-approximate (s,t)-mincut, and its
value is wr(u,v).

In our analysis, we use the notion of a minimal Gomory-Hu tree. We define this next.

Definition 4.4.3: Rooted minimal Gomory-Hu Steiner tree

Given a graph G = (V, E') and a set of terminals U C V', a rooted minimal Gomory-Hu
Steiner tree is a Gomory-Hu Steiner tree on U, rooted at some vertex r € U, with the
following additional property:

() For all t € U\ {r}, consider the minimum-weight edge (u,v) on the unique r —¢
path in T if there are multiple minimum weight edges, let (u,v) denote the
one that is closest to t. Let U’ be the vertices of the connected component of
T — (u,v) containing r. Then, dgf~'(U’) C V is a minimal (r,t)-mincut, and
its value is wr(u,v).

The following theorem establishes the existence of a rooted minimal Gomory-Hu Steiner
tree rooted at any given vertex.
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Theorem 4.4.4: Existence of rooted minimal Gomory-Hu Steiner tree

For any graph G = (V| E), terminals U C V| and root r € U, there exists a rooted
minimal Gomory-Hu Steiner tree rooted at 7.

Proof. Let € > 0 be a small enough weight, and let G’ be the graph G with an additional
edge (r,v) of weight e added for each v € V' \ {r}. (If the edge (r,v) already exists in G,
then increase its weight by € instead.) If € > 0 is small enough, then for all ¢ € V'\ {r} and
S CV,if O¢S is an (r,t)-mincut in G’, then 9¢S is an (r,t)-mincut in G.

Let (77, f) be a Gomory-Hu Steiner tree for G’. We claim that it is essentially a minimal
Gomory-Hu Steiner tree for GG, except that its edge weights need to be recomputed as mincuts
in G and not G'. More formally, let T" be the tree 7" with the following edge re-weighting:
for each edge (u,v) in T, take a connected component U’ of T' — (u,v) and reset the edge
weight of (u,v) to be w(dgf~1(U")) and not w(dg f~1(U")). We now claim that (T, f) is a
minimal Steiner Gomory-Hu tree for G.

We first show that (7, f) is a Gomory-Hu Steiner tree for G. Fix s,t € U, let (u,v) be
the minimum-weight edge on the s—t path in 7", and let U’ be the vertices of the connected
component of 7" — (u,v) containing s. Since (7", f) is a Gomory-Hu Steiner tree for G,
we have that dg f~1(U’) is an (s,t)-mincut in G’. If € > 0 is small enough, then by our
argument from before, dg f~(U’) is also an (s,t)-mincut in G. By our edge re-weighting of
T, the edge (u,v) has the correct weight. Moreover, (u,v) is the minimum-weight edge on
the s—t path in T, since a smaller weight edge would contradict the fact that dg f~1(U’) is
an (s,t)-mincut.

We now show the additional property (x) that makes (7', f) a minimal Gomory-Hu Steiner
tree. Fix t € U \ {r}, and let (u,v) and U’ be defined as in (%), i.e., (u,v) is the minimum-
weight edge (u,v) on the r —¢ path that is closest to ¢, and U’ is the vertices of the connected
component of T'— (u,v) containing . Since (7, f) is a Gomory-Hu Steiner tree for G, we
have that dgf~'(U’) is an (r,t)-mincut of value wy(u,v). Suppose for contradiction that
Oaf~HU") is not a minimal (r,t)-mincut. Then, there exists S C f~1(U’) such that 99 is
also an (r,t)-mincut. By construction of G/, w(9zS) = w(9gS) + |S|e and w(de f~H(U")) =
w(Oaf~HU") + |fH(U")]e. We have w(9gS) = w(dqf(U")) and |S| < |f~1(U")], so
w(0eS) < w(dg f~H(U")). In other words, f~(U’) is not an (r, t)-mincut in G’, contradicting
the fact that (77, f) is a Gomory-Hu Steiner tree for G’. Therefore, property (x) is satisfied,
concluding the proof. m

4.5 Reducing to SSMC Verification

In this section, we prove Theorem The GH tree algorithm is described in Algo-
rithm a few pages down.
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4.5.1 A Single Recursive Step

Before we present Algorithm [GHTREE] we first consider the subprocedure [GHTREESTEP]
that it uses on each recursive step.

Algorithm 2 [GHTREESTEP|G = (V, E),s,U)
1. Initialize R® <~ U and D < ()
2. For all i from 0 to |lg|U|| do:

(a) Call Lemma on T = R', obtaining disjoint sets S? (the minimal (v, R* \ v)-
mincut) for each v € R'.

(b) Call single-source mincut verification on graph G, source s, and values Ay =
w(0S?) for v € R'. (We do not care about any v ¢ R’, so we can set \, = oo for
them.)

(¢) Let D' C R’ be the union of S!NU over all v € R\ {s} satisfying A, = mincut(s, v)
and |S! NU| < |U|/2

(d) R™! < subsample of R’ where each vertex in R’ \ {s} is sampled independently
with probability 1/2, and s is sampled with probability 1

3. Return the largest set D’ and the corresponding sets S over all v € R*\ {s} satisfying
the conditions in line 2d

Let D = D°U D' U --- U DIVl be the union of the sets D' as defined in Algo-
rithm (GHTREESTEP|, Let D* be all vertices v € U \ {s} for which there exists an (s,v)-
mincut whose v side has at most |U]|/2 vertices in U. We now claim that D covers a large
fraction of vertices in D* in expectation.

Lemma 4.5.1: D covers a large fraction of D* in expectation

E[|D 1 D*|] = (|D*|/log |U])-

Proof. Consider a rooted minimal Steiner Gomory-Hu tree T" of G on terminals U rooted at
s, which exists by Theorem [4.4.4 For each vertex v € U \ {s}, let r(v) be defined as the
child vertex of the lowest weight edge on the path from v to s in T. If there are multiple
lowest weight edges, choose the one with the maximum depth.

For each vertex v € D*, consider the subtree rooted at v, define U, to be the vertices in
the subtree, and define n, as the number of vertices in the subtree. We say that a vertex
v € D* is active if v € R' for i = |lgn,(,)). In addition, if U, N R" = {v}, then we say
that v hits all of the vertices in U, (,) (including itself); see Figure . In particular, in order
for v to hit any other vertex, it must be active. For completeness, we say that any vertex in
U\ D* is not active and does not hit any vertex.

To prove that E[|D|] > Q(|D*|/log|U|), we will show that

(a) each vertex u that is hit is in D,
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Figure 4.1: Let i = |lgn,)] = |1g7] = 2, and let the red vertices be those sampled in R*.
Vertex v is active and hits u because v is the only vertex in U, that is red.

(b) the total number of pairs (u,v) for which v € D* hits u is at least ¢|D*| in expectation
for some small enough constant ¢ > 0, and

(c) with probability at least 1 — ST (for the constant ¢ > 0 in (b)), each vertex w is hit
by at most O(log |U|) vertices v € D*.

For (a), consider the vertex v that hits u. By definition, for i = [lgn, )|, we have
UrwyNR" = {v}, s0 0f 1 (Uy()) is a (v, R"\ {v})-cut. By the definition of 7(v), we have that
Of Y (Uyw) is a (v, s)-mincut. On the other hand, we have that 95} is a (v, R"\ {v})-mincut,
so in particular, it is a (v, s)-cut. It follows that 9f ' (U,(,)) and 95! are both (v, s)-mincuts
and (v, R" \ v)-mincuts, and w(9S’) = mincut(s,v) < W. Since T is a minimal Gomory-Hu
Steiner tree, we must have f~(U,(,)) € S.. Since S! is the minimal (v, R* \ {v})-mincut, it
is also the minimal (v, s)-mincut, so S; C f~ (U, (). It follows that f~1(U,(,) = S!. Since
7 (Urvy) is the minimal (v, s)-mincut and v € D*, we must have | f~}(U,,)) NU| < z, so in
particular, |S:NU| = | f~H(U,@u)) NU| < z. Therefore, the vertex v satisfies all the conditions
of line . Moreover, since u € U,y C f’l(Ur(U)) = 5! vertex u is added to D in the set
SinU.

For (b), for i = |lgn, |, we have v € R" with probability exactly 1/2"° = ©(1/n,(,)),
and with probability ©(1), no other vertex in U,(,) joins Ri. Therefore, v is active with
probability €(1/n,)). Conditioned on v being active, it hits exactly n,(,) many vertices. It
follows that v hits Q(1) vertices in expectation.

For (c), the number of vertices v that hit vertex u is at most the number of active vertices
v for which 7(v) is on the path from u to s in 7. Label these vertices u = vy, vy, ..., v, = s,
ordered by increasing distance from u to r(v;) in 7. Each vertex v; € D* is active with
probability ©(1/n,;)), which is at most ©(1/5) since vy, ..., v; € Uy(v,;). Each vertex v; ¢ D*
is never active. Therefore, the expected number of active vertices on the path from u to s
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is at most 25:1 O(1/7) =0(Inf) < O(In|U|). A standard Chernoff bound shows that with
probability at least 1 — 2‘% for any constant ¢ > 0, the number of active vertices on the
path is indeed O(In |U]), where the O(-) hides the dependency on c¢. Taking a union bound
over all u € U, the probability that this is true for all vertices is at least 1 — mﬁ

Finally, we show why properties (a) to (c) imply E[|D|] > Q(|D*|/log|U|). In the event
that property (c) fails, the total number of pairs (u, v) for which v hits u can be trivially upper
bounded by |U|?. Since this occurs with probability at most 2\%’ the total contribution to
the expectation ¢|D*| in property (b) is at most ¢/2. Therefore, the contribution to the
expectation in the event that property (c) succeeds is at least ¢|D*| —¢/2 > (¢/2)|D*|. In
this case, since each vertex is hit at most O(log |U|) times, there are at least Q(|D*|/log |U])

vertices hit in expectation. ]

The corollary below immediately follows. Note that the sets S’ output by the algorithm
are disjoint, which we require in the recursive GH tree algorithm.

Corollary 4.5.2: The largest D’ covers a large fraction of D* in expectation

The largest set D’ returned by(GHTREESTEP|satisfies E[| D'ND*|] = Q(|D*|/ log® |U|).

4.5.2 The Gomory-Hu Tree Algorithm

The Gomory-Hu tree algorithm is presented in which uses [GHTREESTEP| as a
subprocedure on each recursive step.

Correctness. Algorithm has the same recursive structure as Gomory and Hu'’s
original algorithm, except that it computes multiple mincuts on each step. Therefore, cor-
rectness of the algorithm follows similarly to their analysis. For completeness, we include it

below.
Lemma 4.5.3: Correctness of Algorithm GHTREE

Algorithm |(GHTREE(G = (V, E),U) outputs a Gomory-Hu Steiner tree.

To prove Lemma we first introduce a helper lemma.

Lemma 4.5.4: Mincuts in Glage and mincuts in Ujage are preserved exactly

For any distinct vertices p,q € Ularge, We have mincutg,, . (p,q) = mincutg(p,q). The
same holds with Uyree and Glaree replaced by U, and G, for any v € Dt.

Proof. Since Garge is a contraction of G, we have mincutg,, . (p, ¢) > mincutg(p, ¢). To show
the reverse inequality, fix any (p, ¢)-mincut in G, and let S be one side of the mincut. We
show that for each v € R', either S? C Sor S C V\S. Assuming this, the cut 95 stays intact
when the sets S;, are contracted to form Glarge, so mincutg,, . (p, ¢) < w(dS) = mincute(p, q).
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Algorithm 3 |GHTREE(G = (V, E),U)

1.
2.

X N oo

s < uniformly random vertex in U

Call (GHTREESTEP|(G, s,U) to obtain D’ and the sets S (so that D' = |JS! NU)

For each set S¢ do: > Construct recursive graphs and apply recursion

(a) Let G, be the graph G with vertices V '\ S contracted to a single vertex x, > S°
are disjoint

(b) Let U, < S:NU
(c) If |U,| > 1, then recursively set (T, f,) <1 GHTREE(G,, U,)

Let Giarge be the graph G with (disjoint) vertex sets S’ contracted to single vertices y,
for all v € D*

Let Upnrge < U\ D’
If |U,| > 1, then recursively set (Tiarge; farge) 1 GHTREE(Glarge, Ularge)

Combine (Targe, flarge) and {(T,, f») : v € D'} into (T, f) according to
Return (7', f)

Algorithm 4 [COMBINE((Tlarge; fiarge) {(To, fo) 1 v € R'})

1:

Construct T' by starting with the disjoint union Tjuee U |

veri Iy and, for each v € R,

adding an edge between f,(x,) € U, and fiarge(Ys) € Ularge 0f weight w(9gS?)

Construct f : V — U by f(v') = flarge(V') if v' € Ularge and f(v') = f,(v') if o' € U, for

some v € R
return (7, f)
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Consider any v € R, and suppose first that v € S. Then, S! N S is still a (v, R*\ v)-cut,
and S’ U S is still a (p,q)-cut. By the submodularity of cuts,

w(065S,) + w(9eS) = w(06(S, U S)) +w(0a(S, N S)).

In particular, S? NS must be a minimum (v, R’ \ v)-cut. Since S¢ is the minimal (v, R’ \ v)-
mincut, it follows that S° NS = S!  or equivalently, S° C S.

Suppose now that v ¢ S. In this case, we can swap p and ¢, and swap S and V' \ S, and
repeat the above argument to get S? C V'\ S.

The argument for U, and G, is identical, and we skip the details. n

Proof (Lemma . We apply induction on |U|. By induction, the recursive outputs
(Narge, frarge) and (75, f,) are Gomory-Hu Steiner trees. By definition, this means that for
all 2,y € Ulage and the minimum-weight edge (u,w’) on the z—y path in Tiage, letting
Ul’arge C Ularge be the vertices of the connected component of Tiage — (4, %') containing x, we
have that flgrlge(U{arge) is an (s,t)-mincut in Gage with value is wp(u,u'). Define U C U
as the vertices of the connected component of 7' — (u,u’) containing z. By construction of
(T, f) (lines and , the set f~1(U’) is simply fI;rlge(Ul’arge) with the vertex zjag replaced
by V'\ large 11 the case that Tjag. € f7HU"). Since Giarge is simply G with all vertices
V' \ S,ge contracted t0 iage, we conclude that we,,,, (0 fimge (Uhge)) = wa(df~1(U")). By
Lemma we have mincutg(z,y) = mincutg,,  (z,y) are equal, so wg(9f~'(U’)) is an
(z,y)-mincut in G. In other words, the Gomory-Hu Steiner tree condition for (7, f) is
satisfied for all 2,y € Ularge- A similar argument handles the case z,y € U, for some v € R

There are two remaining cases: x € U, and y € Ularge, and x € U, and y € U,y for distinct
v,v" € R'. Suppose first that € U, and y € Ularge- By considering which sides v and s lie

on the (z,y)-mincut, we have
w(0gS) = mincut(z, y) > min{mincut(z, v), mincut(v, s), mincut(s, y)}.

We now case on which of the three mincut values mincut(z,y) is greater than or equal to.

1. If mincut(z,y) > mincut(v, s), then since S’ is a (v, s)-mincut that is also an (z, y)-cut,
we have mincut(x,y) = mincut(v, s). By construction, the edge (f,(zy), fiarge(Yn)) Of
weight w(0gS?) = w(9gS) is on the x — y path in T. There cannot be edges on the
x —t path in T of smaller weight, since each edge corresponds to a (s,t)-cut in G of
the same weight. Therefore, (f,(2,), flarge(y»)) is the minimum-weight edge on the s—¢
path in T

2. Suppose now that mincut(z,v) < mincut(z,y) < mincut(v, s). The minimum-weight
edge e on the z — v path in T, has weight mincut(z,v). This edge e cannot be on the
v— fy(z,) path in T, since otherwise, we would obtain a (v, z,)-cut of value mincut(zx, v)
in GG,, which becomes a (v, s)-cut in G after expanding the contracted vertex z,; this
contradicts our assumption that mincut(z,v) < mincut(v, s). It follows that e is on the
x — fy(x,) path in T, which, by construction, is also on the x — y path in 7. Once
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again, the x — y path cannot contain an edge of smaller weight.
3. The final case mincut(s,y) < mincut(z,y) < mincut(v, s) is symmetric to case except
we argue on Tiaree and Giage instead of T, and G,,.
Suppose now that z € U, and y € U, for distinct v,v" € R'. By considering which sides
v,v', s lie on the (z,y)-mincut, we have

w(9gS) = mincut(x, y) > min{mincut(x,v), mincut(v, s), mincut(s, v"), mincut(v’, y) }.

We now case on which of the four mincut values mincut(x,y) is greater than or equal to.
1. If mincut(x,y) > mincut(v, s) or mincut(z,y) > mincut(s, v), then the argument is the
same as case [l above.
2. If mincut(z,v) < mincut(z,y) < mincut(v,s) or mincut(y,v’) < mincut(x,y)
< mincut(v', s), then the argument is the same as case [2| above.
This concludes all cases, and hence the proof. O

Lemma 4.5.5: Recursion depth

W.h.p., the algorithm has maximum recursion depth O(log® n).

Proof. By construction, each recursive instance (G,,U,) has |U,| < |U|/2. We use the
following lemma from [3].

Lemma 4.5.6: Random selection of s [3]

Suppose the source vertex s € U is chosen uniformly at random. Then, E[|D*|] =
QU -1).

By Corollary and Lemma [4.5.6, over the randomness of s and [GHTREESTEP], we have

E[D'] > Q(E[|D*[]/log® [U]) > Q((|U| — 1)/ log” |U]),

so the recursive instance (Giarge, Ularge) satisfies E[|Uage|] < (1 — 1/log? |U]) - (|U] — 1).
Therefore, each recursive branch either has at most half the vertices in U, or has at most
a (1 —1/log?|U|) fraction in expectation. It follows that w.h.p., all branches terminate by
O(log® n) recursive calls. O

Lemma 4.5.7: Running time

For an unweighted /weighted graph G = (V| E'), and terminals U C V| |GHTREE(G, V)
takes time O(m) plus calls to max-flow on unweighted /weighted instances with a total
of O(n) vertices and O(m) edges.

Proof. For a given recursion level, consider the instances {(G;, U;, W;)} across that level. By
construction, the terminals U; partition U. Moreover, the total number of vertices over all
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G, is at most n + 2(|JU| — 1) = O(n) since each branch creates 2 new vertices and there are
at most |U| — 1 branches.

To bound the total number of edges, we consider the unweighted and weighted cases
separately, starting with the unweighted case. The total number of new edges created is at
most the sum of weights of the edges in the final (1 + €)-approximate Gomory-Hu Steiner
tree. For an unweighted graph, this is O(m) by the following well-known argument. Root
the Gomory-Hu Steiner tree T" at any vertex r € U; for any v € U \ r with parent u, the cut
J{v} in G is a (u,v)-cut of value deg(v), so wr(u,v) < deg(v). Overall, the sum of the edge
weights in 7" is at most ) ., deg(v) < 2m.

For the weighted case, define a parent vertex in an instance as a vertex resulting from
either (1) contracting V'\ S? in some previous recursive G, call, or (2) contracting a compo-
nent containing a parent vertex in some previous recursive call. There are at most O(logn)
parent vertices: at most O(logn) can be created by (1) since each G, call decreases |U| by a
constant factor, and (2) cannot increase the number of parent vertices. Therefore, the total
number of edges adjacent to parent vertices is at most O(logn) times the number of vertices.
Since there are O(n) vertices in a given recursion level, the total number of edges adjacent to
parent vertices is O(nlogn) in this level. Next, we bound the number of edges not adjacent
to a parent vertex by m. To do so, we first show that on each instance, the total number
of these edges over all recursive calls produced by this instance is at most the total number
of such edges in this instance. Let P C V be the parent vertices; then, each G, call has
exactly |E(G[S? \ P])| edges not adjacent to parent vertices (in the recursive instance), and
the Glarge call has at most |E(G[V'\ P])\ U, E(G[S%\ P])|, and these sum to |[E(G[V \ P])|,
as promised. This implies that the total number of edges not adjacent to a parent vertex at
the next level is at most the total number at the previous level. Since the total number at
the first level is m, the bound follows.

Therefore, there are O(n) vertices and O(m) edges in each recursion level. By Lemma ,
there are O(e ' log® n) levels (since A = 1 for an unweighted graph), for a total of O(ne )
vertices and O(me ') edges. In particular, the instances to the max-flow calls have O(ne ™)
vertices and O(me™') edges in total. O

4.6 The Cut Threshold Algorithm

We now present the cut threshold (CT) algorithm, an important building block for the
approximate GH tree algorithm. We first describe a single step of the [CUTTHRESHOLD|
algorithm (we call this (CUTTHRESHOLDSTEP)).

We remark that throughout this section, we will always set 2z = 00, so the constraint
|S: N U| < z in line 4| can be ignored. However, the variable z will play a role in the next
section on computing a Gomory-Hu tree.

Let D = D°U D' U---U DUVl be the union of the sets output by the algorithm. Let
D* be all vertices v € U \ s for which there exists an (s, v)-cut of weight at most W whose
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Algorithm 5 [CUTTHRESHOLDSTEP(G = (V, E),s, U, W, 2)
1: Initialize R® <~ U and D « ()
2: for i from 0 to |lg|U|] do

3:  Compute minimum isolating cuts {S! : v € R’} on inputs G and R’

4:  Let D' be the union of S° N U over all v € R"\ {s} satisfying w(dS!) < W and
1SENU| <z

5 R « subsample of R’ where each vertex in R*\ {s} is sampled independently with

probability 1/2, and s is sampled with probability 1
6: return D°U D' U ..U DUl

side containing v has at most z vertices in U.
The lemma below is similar to Lemma [£.5.1] and their proofs share a lot of overlap.

Lemma 4.6.1: D covers a large fraction of D* in expectation

D C D* and E[|D|] = Q(|D*|/log |U]).

Proof. We first prove that D C D*. Each vertex v € D belongs to some S satisfying
w(90S?) < W and |S! NU| < z. In particular, 0S¢ is an (s, u)-cut with weight at most W
whose side S? containing u has at most z vertices in U, so u € D*.

It remains to prove that E[|D|] > Q(]D*|/log|U]|). Consider a rooted minimal Steiner
Gomory-Hu tree T of G on terminals U rooted at s, which exists by Theorem [£.4.4] For
each vertex v € U \ {s}, let r(v) be defined as the child vertex of the lowest weight edge on
the path from v to s in T'. If there are multiple lowest weight edges, choose the one with the
maximum depth.

For each vertex v € D*, consider the subtree rooted at v, define U, C D* to be the
vertices in the subtree, and define n, as the number of vertices in the subtree. We say that
a vertex v € D* is active if v € R’ for i = [lgn, ). In addition, if Uy, N R = {v},
then we say that v hits all of the vertices in U, (including itself); see Figure again. In
particular, in order for v to hit any other vertex, it must be active. For completeness, we
say that any vertex in U \ D* is not active and does not hit any vertex.

To prove that E[|D|] > Q(|D*|/log|U|), we will show that

(a) each vertex u that is hit is in D,

(b) the total number of pairs (u,v) for which v € D* hits u is at least ¢|D*| in expectation
for some small enough constant ¢ > 0, and

(c) with probability at least 1 — ST (for the constant ¢ > 0 in (b)), each vertex w is hit
by at most O(log |U|) vertices v € D*.

For (a), consider the path from u to the root s in 7', and take any vertex v € D* on
the path that is active (possibly w itself). Such a vertex must exist since w is hit by some
vertex. By definition, for i = |lgn,@) ], we have U,y N R = {v}, so f HU,w) is a
(v, R"\ {v})-cut. By the definition of r(v), we have that df~(U,(,) is a (v, s)-mincut. On
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the other hand, we have that 9S! is a (v, R* \ {v})-mincut, so in particular, it is a (v, s)-
cut. It follows that 0f ' (U,() and 9S. are both (v, s)-mincuts and (v, R'\ v)-mincuts,
and w(9S!) = mincut(s,v) < W. Since T is a minimal Gomory-Hu Steiner tree, we must
have f~'(U,)) C Si. Since S¢ is the minimal (v, R* \ {v})-mincut, it is also the minimal
(v, s)-mincut, so S; C f~1(U,q)). It follows that f~'(U,q)) = Si. Since f~(U,)) is the
minimal (v, s)-mincut and v € D*, we must have |f~(U,)) N U| < z, so in particular,
1SiNU| = |f~Y(Uyw)) NU| < z. Therefore, the vertex v satisfies all the conditions of line .
Moreover, since u € U,y C f~HUpw)) = SP, vertex u is added to D in the set S; N U.

For (b), for i = |lgn,)|, we have v € R" with probability exactly 1/2° = ©(1/n,(,)),
and with probability ©(1), no other vertex in U, joins Ri. Therefore, v is active with
probability ©(1/n,¢)). Conditioned on v being active, it hits exactly n,.,) many vertices. It
follows that v hits (1) vertices in expectation.

For (c), the number of vertices v that hit vertex u is at most the number of active vertices
v for which 7(v) is on the path from u to s in 7. Label these vertices u = vy, vy, ..., v, = s,
ordered by increasing distance from u to r(v;) in 7. Each vertex v; € D* is active with
probability ©(1/n,;)), which is at most ©(1/5) since vy, ..., v; € Uy(v,). Each vertex v; ¢ D*
is never active. Therefore, the expected number of active vertices on the path from u to s
is at most 25:1 O(1/7) =0(In¢) < O(In|U|). A standard Chernoff bound shows that with
probability at least 1 — ﬁ for any constant ¢ > 0, the number of active vertices on the
path is indeed O(In |U]), where the O(-) hides the dependency on c¢. Taking a union bound
over all u € U, the probability that this is true for all vertices is at least 1 — ﬁ

Finally, we show why properties (a) to (c) imply
E[|D|] > Q(|D*|/log|U|). In the event that property (c) fails, the total number of pairs
(u,v) for which v hits u can be trivially upper bounded by |U|*. Since this occurs with
probability at most ﬁ, the total contribution to the expectation ¢|D*| in property (b) is
at most ¢/2. Therefore, the contribution to the expectation in the event that property (c)
succeeds is at least ¢|D*| — ¢/2 > (¢/2)|D*|. In this case, since each vertex is hit at most
O(log |U|) times, there are at least Q(|D*|/log|U|) vertices hit in expectation, all of which
are included in D by property (a). ]

We now use iterate Algorithm [CUTTHRESHOLDSTEP| to obtain the [CUTTHRESHOLD|
algorithm:

Algorithm 6 |[CUTTHRESHOLD(G = (V. E), s, W)
1: Initialize U < V and Dyga + 0
2: for O(log®n) iterations do
3: Let D be the union of the sets output by |[CUTTHRESHOLDSTEP|G, s, U, W, 00)
4
5

Update Diotal < Dot U D and U < U \ D

: return Digig
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Corollary 4.6.2: Correctness of CUTTHRESHOLD

W.h.p., the output Diua of [CUTTHRESHOLD] is exactly all vertices v € U \ {s} for
which the (s, v)-mincut has weight at most W.

Proof. By Lemma |UN D*| decreases by Q(|D*|/logn) in expectation. After O(log®n)
iterations, we have E[|U N D*|] < 1/poly(n), so w.h.p., U N D* = (). Each vertex in D* that
is removed from U is added to Diota1, and no vertices in U \ D* are added to Dygtal, 50 w.h.p.,
the algorithm returns the correct set D*. [

In other words, [CUTTHRESHOLD] is an algorithm that fulfills Theorem [4.2.8

4.7 Approximate GH Tree

Let € > 0 be a fixed parameter throughout the recursive algorithm. We present our approxi-
mate Steiner Gomory-Hu tree algorithm in [APPROXSTEINERGHTREE| below. See Figure(4.2
for a visual guide to the algorithm.

At a high level, the algorithm applies divide-and-conquer by cutting the graph along
sets S! computed by [CUTTHRESHOLDSTEP|, applying recursion to each piece, and stitching
the recursive Gomory-Hu trees together in the same way as the standard recursive Gomory-
Hu tree construction. To avoid complications, we only select sets S? from a single level
i€{0,1,2...,[lg|U|]}, which are guaranteed to be vertex-disjoint. Furthermore, instead of
selecting all sets {S? : v € R}, we only select those for which |S!NU| < |U|/2; this allows us
to bound the recursion depth. By choosing the source s € U at random, we guarantee that
in expectation, we do not exclude too many sets S?. The chosen sets partition the graph into
disjoint sets of vertices (including the set of vertices outside of any chosen set S?). We split
the graph along this partition a similar way to the standard Gomory-Hu tree construction:
for each set in the partition, contract all other vertices into a single vertex and recursively
compute the Steiner Gomory-Hu tree of the contracted graph. This gives us a collection of
Gomory-Hu Steiner trees, which we then stitch together into a single Gomory-Hu Steiner
tree in the standard way.

4.7.1 Approximation

Since the approximation factors can potentially add up down the recursion tree, we need
to bound the depth of the recursive algorithm. Here, there are two types of recursion: the
recursive calls (G, U, ), and the single call (Giarge, Ularge). Taking a branch down (G, U,) is
easy: since |U,| < |U|/2, the algorithm can travel down such a branch at most lg |U| times.
The difficult part is in bounding the number of branches down (Glarge, Ularge). It turns out
that after polylog(n) consecutive branches down (Glarge; Ularge), the Steiner mincut increases
by factor (1 + €), w.h.p.; we elaborate on this insight in Section [.7.2] which concerns the
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Figure 4.2: Recursive construction of Giae and G, for v € R Here, R. ., = {v1,v2,v3},
denoted by red vertices on the top left. The dotted blue curves on the right mark the
boundaries of the regions f,'(u) : u € U,, and ( ) : 4 € Ularge- The light green edges

on the bottom left are the edges (f.,(zy,), flarge(yvl)) added on line [1] of [COMBINE]

small *

Algorithm 7 |[APPROXSTEINERGHTREE(G = (V, E),U)

1: X\ < global Steiner mincut of G with terminals U

2: s < uniformly random vertex in U

3: Call|CUTTHRESHOLDSTEP(G, s, U, (1+¢€)A, |U|/2), and let R/ and S? :v € R/ (0 < j <
lg |U|) be the intermediate variables in the algorithm

4: Let i € {0,1,..., [1g|U|]} be the iteration maximizing | |J

vERZ SZ N U)‘

5: for each v € R* do > Construct recursive graphs and apply recursion

6: Let G, be the graph G with vertices V'\ S? contracted to a single vertex z, > S! are
disjoint

7. LetU,+ S'NU

8: (Ty, fv) < APPROXSTEINERGHTREE(G,, U,)

9: Let Glarge be the graph G with (disjoint) vertex sets S¢ contracted to single vertices y,
for all v € R

10: Let Uyarge <= U\ Uyepi (S5 NU)

11: (Targes fiarge) ${ APPROXSTEINERGHTREE(Glarge; Ularge)

12: Combine (Tiarge, flarge) and {(Ty, f,) : v € R'} into (T, f) according to
13: return (7, f)
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running time. Since the Steiner mincut can never decrease down any recursive branch, it
can increase by factor (1 + €) at most e 'polylog(n)log A times. Thus, we have a bound of
¢ 'polylog(n) log A on the recursion depth, w.h.p.

This depth bound alone is not enough for the following reason: if the approximation
factor increase by (14 €) along each recursive branch, then the total approximation becomes
(1 4 )¢ 'pobvlog(mlosA " which is no good because the (1 4 ¢) and €' cancel each other.
Here, our key insight is that actually, the approximation factor does not distort at all down
(Glarge, Utarge)- It may increase by factor (1+€) down any (G, U,), but this can only happen
lg |U| times, giving us an approximation factor of (1 4 ¢)'¢!Y!, which is fine because we can
always retroactively replace ¢ with ©(e/lg|U|) to obtain the desired (1 + €).

The lemma below formalizes our insight that approximation factors are preserved down

the branch (Glarge, Ularge)-

Lemma 4.7.1: Mincuts in Gage are preserved exactly

For any distinct vertices D, q € vz we have
mincutg,,.. (p, q) = mincutg(p, q).

Proof. Since Glarge is a contraction of G, we have
mincutg,, . (p,q) > mincutg(p,q). To show the reverse inequality, fix any (p,¢)-mincut in
G, and let S be one side of the mincut. We show that for each v € R’, either S} C S or
S{ C V'\ S. Assuming this, the cut 95 stays intact when the sets S? are contracted to form
Glarge, 80 mincutg,,  (p,q) < w(9S) = mincutg(p, q).

Consider any v € R’, and suppose first that v € S. Then, S! N S is still a (v, R* \ v)-cut,
and S’ U S is still a (p, ¢)-cut. By the submodularity of cuts,

w(06S1) + w(96S) > w(de(St U S)) + w(da(SiN S)).

In particular, S! NS must be a minimum (v, R* \ v)-cut. Since S? is the minimal (v, R*\ v)-
mincut, it follows that S’ NS = S!, or equivalently, S’ C S.

Suppose now that v ¢ S. In this case, we can swap p and ¢, and swap S and V' \ S, and
repeat the above argument to get S? C V'\ S. n

Similarly, the lemma below says that approximation factors distort by at most (1 + €)
down a (G, U,) branch.

Lemma 4.7.2: Mincuts in G, are preserved (1 + €)-approximately

For any v € R' and any distinct vertices p,q € U,, we have mincutg(p,q) <
mincutg, (p, ¢) < (1 + €)mincutg(p, q).

Proof. The lower bound mincuts(p, ¢) < mincutg, (p, ¢) holds because G, is a contraction of
G, so we focus on the upper bound. Fix any (p,q)-mincut in G, and let S be the side of
the mincut not containing s (recall that s € U and s ¢ S!). Since S U S is a (p, s)-cut
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(it is also a (g, s)-cut), it is in particular a Steiner cut for terminals U, so w(S? U S) > .
Also, w(S%) < (1+ €)X by the choice of the threshold (1 + €)A (line [3). Together with the
submodularity of cuts, we obtain

(1 + 6))\ + w(@GS) > w(@GS;) + w(@GS)
> w(06(S, U S)) +w(d6(S,NS))
> A

+w(96(S1 N S)).

The set S! NS stays intact under the contraction from G to G,, so w(dg, (S N S)) =
w(9g(S: N S)). Therefore,

mincutg, (p, ¢) < w(dg, (S, N S))
= w(9g(S, N S))
< w(06S) + €A
< mincutg(p, q) + € mincute(p, q)
as promised. -

Finally, the lemma below determines our final approximation factor.

Lemma 4.7.3: Approximation factor

IAPPROXSTEINERGHTREE(G = (V, E), U) outputs a (1+¢)'¢!Vl-approximate Gomory-
Hu Steiner tree.

Proof. We apply induction on |U]|. Since |U,| < |U]/2 for all v € R, by induction, the
recursive outputs (75, f,) are Gomory-Hu Steiner trees with approximation (1 4 €)%/ <
(14 ¢)'lUI=1 By definition, this means that for all s,* € U, and the minimum-weight edge
(u,u’) on the s—t path in T, letting U, C U, be the vertices of the connected component of
T, — (u,u') containing s, we have that f;1(U!) is a (1 + €)®8IVI=L_approximate (s, t)-mincut
in G, with value is wr(u,u’). Define U' C U as the vertices of the connected component of
T — (u,u’) containing s. By construction of (T, f) (lines [l and [2), the set f~(U’) is simply
1,7 H(U!) with the vertex z,, replaced by V\S! in the case that x, € f~1(U’). Since G, is simply
G with all vertices V'\ S! contracted to z,,, we conclude that wg, (f, *(U!)) = wg(df~H(U")).
By Lemma[4.7.2] the values mincutg(s, ) and mincutg, (s, ) are within factor (1 +€) of each
other, so wg(0f~H(U")) approximates the (s, t)-mincut in G to a factor (14-¢)-(14¢)®lVI=1 =
(1 + ¢)'8lUl. In other words, the Gomory-Hu Steiner tree condition for (7, f) is satisfied for
all 5,t € U, for some v € R'.

By induction, the recursive output (Tjarge, fiarge) is @ Gomory-Hu Steiner tree with approx-
imation (1 + €)8Parsel < (1 4 €)'81Vl. Again, consider s, € Ujarge and the minimum-weight
edge (u,u’) on the s—t path in Tiage, and let Ul’arge C Ularge be the vertices of the connected
component of Tjaee — (u, u') containing s. Define U’ C U as the vertices of the connected com-
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ponent of 7" — (u, ') containing s. By a similar argument, we have wg,,,, (0 flgrlge(U{arge)) =
we(0f~HU)). By Lemma [4.7.] we also have
mincutc (s, t) = mincutg,,,,. (s, t), so wa(0f~H(U’)) is a (1 + €)' !Vl-approximate (s, ¢)-mincut
in G, fulfilling the Gomory-Hu Steiner tree condition for (7, f) in the case s,t € Ularge.
There are two remaining cases: s € U, and t € U, for distinct v,v" € R?, and s € U,
and t € Uarge; We treat both cases simultaneously. Since G has Steiner mincut A, each of
the contracted graphs Giage and G, has Steiner mincut at least A. By induction, every
edge in T, and Tjage or T,y (depending on case) has weight at least (1 + €)"18lUIN. By
construction, the s—¢ path in 7" has at least one edge of the form (f,(z,), flarge(y»)), added
on line [1} this edge has weight w(9gS?) < (1+ €)\. Therefore, the minimum-weight edge on
the s—t path in T has weight at least (1 4+ ¢)~'8YI\ and at most (1 + €)); in particular, it
is a (1 + €)'lVLapproximation of mincutg(s, ). If the edge is of the form (f,(2,), fiarge(Ys)),
then by construction, the relevant set f~!(U’) is exactly S!, which is a (1 + ¢)-approximate
(s,t)-mincut in G. If the edge is in Tjapge Or T}, or T}y, then we can apply the same arguments
used previously. O]

4.7.2 Running Time Bound

In order for a recursive algorithm to be efficient, it must make substantial progress on each
of its recursive calls, which can then be used to bound its depth. For each recursive call
(Gy, Uy, €), we have |U,| < |U|/2 by construction, so we can set our measure of progress to
be |U|, the number of terminals, which halves upon each recursive call. However, progress
on (Giarge; Ularge, €) 1s unclear; in particular, it is possible for |Ujage| to be very close to |U]
with probability 1. For Giage, we define the following alternative measure of progress. Let
P(G,U,W) be the set of unordered pairs of distinct vertices whose mincut is at most W:

PG UW) = {{u,v} e (g) - mincutg (u, v) < W}.

In particular, we will consider its size |P(G, U, W)|, and show the following expected reduc-
tion:

Lemma 4.7.4: Expected reduction of |P(G,U, W)| in (Glarge, Utarge)

For any W < (1 + €)A, over the random selection of s and the randomness in
[THRESHOLDSTEP|, we have

B[P Guws U W < (1-0 (= ) ) 1P(G. 0 )L

log”n

. J

Before we prove Lemma [4.7.4] we show how it implies progress on the recursive call for

Glarge .
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Corollary 4.7.5:

Let g be the global Steiner mincut of G. W.h.p., after Q(log® n) recursive calls along
Glarge (replacing G <— Giage €ach time), the global Steiner mincut of G is at least
(14 €)Ao (where )\ is still the global Steiner mincut of the initial graph).

Proof. Let W = (1 4+ €)Xo. Initially, we  trivially  have
|P(G,UW)| < ('gl). The global Steiner mincut can only increase in the recursive calls,
since Glarge is always a contraction of G, so we always have W < (1 + €)\ for the current
global Steiner mincut A. By Lemma(4.7.4] the value |P(G, U, W)| drops by factor 1 —Q(ﬁ)

n

in expectation on each recursive call, so after Q(log3 n) calls, we have

ancrns (4). (-o(oh))™ "

In other words, w.h.p., we have |P(G,U,W)| = 0 at the end, or equivalently, the Steiner
mincut of G is at least (1 4 €)\. O

Combining both recursive measures of progress together, we obtain the following bound
on the recursion depth:

Lemma 4.7.6: Recursion depth bound of APPROXSTEINERGHTREE

Let wpny, and wpae be the minimum weight and maximum weight of any edge
in G. W.h.p., the depth of the recursion tree of |[APPROXSTEINERGH'IREE] is
O(e'log® nlog(nA)).

Proof. For any O(log®n) successive recursive calls down the recursion tree, either one call
was on a graph G,, or O(log’ n) of them were on the graph Glage. In the former case, |U]
drops by half, so it can happen O(logn) times total. In the latter case, by Corollary ,
the global Steiner mincut increases by factor (1 4 €). Let Wy, and wpax be the minimum
and maximum weights in G, so that A = Wyax/Wmin. Note that for any recursive instance
(G',U’) and any s,t € U’, we have wp, < mincute(s,t) < w(0({s})) < nwmpax, so the global
Steiner mincut of (G’,U’) is always in the range [Wyin, MWmax]. It follows that calling Garge
can happen O(e™! log(nwmax/Wmin)) times, hence the bound. O

We state the next theorem for unweighted graphs only. For weighted graphs, there is
no nice bound on the number of new edges created throughout the algorithm, and therefore
no easy bound on the overall running time. In the next section, we introduce a graph
sparsification step to handle this issue.
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Lemma 4.7.7: Running time bound of APPROXSTEINERGHTREE, unweighted graphs

only

For an wnweighted graph G = (V,E), and terminals U C V|

J

IAPPROXSTEINERGHTREE|(G, V,¢) takes time O(me ') plus calls to max-flow
on instances with a total of O(ne™!) vertices and O(me™!) edges.

Proof. For a given recursion level, consider the instances
{(G;,U;,W;)} across that level. By construction, the terminals U; partition U. Moreover,
the total number of vertices over all G; is at most n 4 2(|U| — 1) = O(n) since each branch
creates 2 new vertices and there are at most |[U| — 1 branches. The total number of new
edges created is at most the sum of weights of the edges in the final (1 + €)-approximate
Gomory-Hu Steiner tree. For an unweighted graph, this is O(m) by the following well-known
argument. Root the Gomory-Hu Steiner tree T" at any vertex r € U; for any v € U \ r with
parent u, the cut d{v} in G is a (u,v)-cut of value deg(v), so wr(u,v) < deg(v). Overall,
the sum of the edge weights in T is at most ) ., deg(v) < 2m.

Therefore, there are O(n) vertices and O(m) edges in each recursion level. By
Lemma m there are O(e 'log’n) levels (since A = 1 for an unweighted graph), for
a total of O(ne™") vertices and O(me ') edges. In particular, the instances to the max-flow
calls have O(ne ') vertices and O(me™!) edges in total. O

Combining Lemmas {4.7.3 and [4.7.7] and resetting ¢ < ©O(¢/logn), we obtain Theo-
rem 4.2.0]

Finally, we prove Lemma [4.7.4]

Proof (Lemmal4.7.4)). Let D* be all vertices v € U \ s for which there exists an (s,v)-cut
of weight at most W whose side containing v has at most |U|/2 vertices in U. Define
D = UJLEJ)UH Uperi (SENU). Let Pogerea(G, U, W) be the set of ordered pairs (u,v) : u,v € V
for which there exists an (u, v)-mincut of weight at most W with at most |U|/2 vertices in U
on the side S(u,v) C V containing u. We now state and prove the following four properties:
(a) For all u,v € U, {u,v} € P(G,U,W) if and only if either (u,v) € Pyderea(G, U, W) or
(v,u) € Porgerea(G, U, W) (or both).
(b) For each pair (u,v) € Podered(G, U, W), we have u € D* with probability at least 1/2,
(c) For each w € D* there are at least |U|/2 vertices v € U for which
(u, U) - Pordered(G; U, W)
(d) Over the randomness in [CUTTHRESHOLDSTEP(G,U, (1  +  €)A),
B[ D[] = Q(|D*|/ log |U).

Property (a) follows by definition. Property (b) follows from the fact that « € D* when-
ever s ¢ S(u,v), which happens with probability at least 1/2. Property (c) follows because
any vertex v € U\ S(u, v) satisfies (u,v) € Pordered (G, U, W), of which there are at least |U|/2.
Property (d) follows from Lemma applied on [CUTTHRESHOLDSTEP(G, U, W, |U|/2),
and then observing that even though we actually call (CUTTHRESHOLDSTEP(G, U, (1 +
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€)A\,|U|/2), the set D can only get larger if the weight parameter is increased from W
to (1 +€)A.

With properties (a) to (d) in hand, we now finish the proof of Lemma[4.7.4] Consider the
iteration ¢ maximizing the size of D" := {J,cp: (S;NU) (line, so that |DY| > |D|/(|1g|U|] +
1). For any vertex u € D', all pairs (u,v) € Porderea(G, U, W) (over all v € U) disappear
from Pyrgerea (G, U, W), which is at least |U|/2 many by (c). In other words,

|Pordered(Ga U7 W) \ Pordered(Glarge> Ulargea W)|

> iy
2

> q(lUl-1DIY
- log [U|

Taking expectations and applying (d),

]E[lpordered(G> U7 W) \ Pordered(Glargea Ularg67 W) H
- o (IVL-ElD]
- log U]

ZQ<\UI'2\D !)'
log” |U|

\U| - |D*| = E[[{(u,v) : u € D*}

Moreover,
1
] 2 §‘Pordered<G7 U7 W)’?

where the second inequality follows by (b). Putting everything together, we obtain

IE“P)ordered(Gy U, W) \ Pordered(Glargea Ularge7 W) H

> Q |Pordered(G7 U, W)| .
- log [U|

Finally, applying (a) gives

E[|P(G,U, W)\ P(Glasge, Vtarge, W)I| = © (M)

log |U|

Finally, we have P(Glarge, Ularge, W) C P(G,U, W) since the (u,v)-mincut for u,v € Uarge
can only increase in Giapge due to Glage being a contraction of G (in fact it says the same by
Lemma |4.7.1]). Therefore,

|P(G, U, W)| - |P(Glarge7 Ulargea W)|
= |P(G, U, W) \ P<Glargea Ulargea W)|7
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and combining with the bound on

E[|P(G,U, W)\ P(Garges Utarge; W)|] concludes the proof. O

4.7.3 Weighted Graphs

For weighted graphs, we cannot easily bound the total size of the recursive instances. Instead,
to keep the sizes of the instances small, we sparsify the recursive instances to have roughly
the same number of edges and vertices. By the proof of Lemma [£.7.7], the total number
of vertices over all instances in a given recursion level is at most n + 2(|U| — 1) = O(n).
Therefore, if each such instance is sparsified, the total number of edges becomes O(n), and
the algorithm is efficient.

It turns out we only need to re-sparsify the graph in two cases: when we branch down
to a graph G, (and not Glarge), and when the mincut A increases by a constant factor, say
2. The former can happen at most O(logn) times down any recursion branch, since |U|
decreases by a factor 2 each time, and the latter occurs O(log(nA)) times down any branch.
Each time, we sparsify up to factor 1 + ©(e/log(nA)), so that the total error along any
branch is 1 + O(e).

We now formalize our arguments. We begin with the specification routine due to Benczur
and Karger [15].

Theorem 4.7.8: Graph sparsification

Given a weighted, undirected graph GG, and parameters €, > 0, there is a randomized
algorithm that with probability at least 1 — § outputs a (1 + €)-approximate sparsifier
of G with O(ne=2log(n/J)) edges.

We now derive approximation and running time bounds.

Theorem 4.7.9: Running time bound of APPROXSTEINERGHTREE, weighted graphs

Suppose that the recursive algorithm [APPROXSTEINERGHT'REE]| sparsifies the input
in the following three cases, using Theorem with the same parameter ¢ and the
parameter 6 = 1/poly(n):

1. The instance was the original input, or
2. The instance was obtained from calling (G, U, ), or

3. The instance was obtained from calling (Giarge, Ularge), and the Steiner mincut
increased by a factor of at least 2 since the last sparsification.
Then w.h.p., the algorithm outputs a (1 +¢)?0°e("A)_approximate Gomory-Hu Steiner
tree and takes O(m) time plus calls to max-flow on instances with a total of
O(ne'log A) vertices and O(ne 'log A) edges.

Proof. We first argue about the approximation factor. Along any branch of the recursion
tree, there is at most one sparsification step of type (1), at most O(logn) sparsification steps
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of type (2), and at most O(log(nA)) sparsification steps of type (3). Each sparsification
distorts the pairwise mincuts by a (1 + €) factor, so the total distortion is (1 + ¢)©os(n&),

Next, we consider the running time. The recursion tree can be broken into chains of
recursive Giage calls, so that each chain begins with either the original instance or some
intermediate G, call, which is sparsified by either (1) or (2). Fix a chain, and let n’ be
the number of vertices at the start of the chain, so that the number of edges is O(n’logn).
Within each chain, the number of vertices can only decrease down the chain. After each
sparsification, many sparsifications of type (2), and between two consecutive sparsifications,
the number of edges can only decrease down the chain since the graph can only contract. It
follows that each instance in the chain has at most n’ vertices and O(n’e 2logn) edges. By
Lemma , each chain has length O(e~'log® nlog(nA)), so the total number of vertices
and edges in the chain is O(n/e ®log A). Imagine charging these vertices and edges to the
n' vertices at the root of the chain. In other words, to bound the total number of edges in
the recursion tree, it suffices to bound the total number of vertices in the original instance
and in intermediate G, calls.

In the recursion tree, there are n original vertices and at most 2(|JU| — 1) new vertices,
since each branch creates 2 new vertices and there are at most |U| — 1 branches. Each vertex
joins O(logn) many G, calls, since every time a vertex joins one, the number of terminals
drops by half; note that a vertex is never duplicated in the recursion tree. It follows that
there are O(nlogn) many vertices in intermediate G, calls, along with the n vertices in the
original instance. Hence, from our charging scheme, we conclude that there are a total of
O(ne?log A) vertices and edges in the recursion tree. In particular, the instances to the
max-flow calls have O(ne3log A) vertices and edges in total. O

Resetting € « O(e/log(nA)), we have thus proved Theorem [£.2.5

4.8 Conclusion

In this chapter, we presented a reduction from exact Gomory-Hu tree to a simpler, seemingly
more tractable problem which we named single-source mincut verification. Although we
could not improve the running time of the latter beyond the trivial n — 1 max-flows, we
showed that improvements could be made in the approximate version. Our main algorithmic
result was an (1 + ¢€)-approximate Gomory-Hu tree in roughly (exact) max-flow time.

Of course, the reduction from approximate Gomory-Hu tree to exact max-flow is un-
satisfactory; a reduction to approximate max-flow would instead lead to a near-linear time
algorithm. However, there is a fundamental barrier to this endeavor: the isolating cuts
lemma (as stated in Lemma does not hold in the approximate setting. Fortunately, we
discovered a variant of the isolating cuts lemma that is robust to approximations, although
it requires more than just computing approximate mincuts. The additional property we
require is that the mincuts are “well-linked” in a sense, and achieving this guarantee is far
from trivial.
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Chapter 5

Directed Global Mincut

In this chapter, we present our algorithm for global mincut on directed graphs based on the
work of [I§]. While a near-linear time algorithm for the undirected global mincut problem
was known since Karger [55], nothing close to linear has been found for directed graphs.
Indeed, Karger’s undirected global mincut algorithm breaks down in multiple ways in the
directed setting. The most glaring issue is the graph sparsification step, namely that directed
graphs are notoriously difficult to sparsify. Nevertheless, in this chapter, we show that under
a locality assumption, a partial sparsification of directed graphs is possible, demonstrating
again how locality can be used to make seemingly impossible problems tractable.

Once again, our specific locality assumption is that the target solution is unbalanced:
one side of the mincut has at most r vertices for some parameter r. In this case, we are able
to sparsify the directed graph so that the original mincut is still approximately a mincut
in the sparsified graph, although the size of the sparsifier is not perfect and depends on
the parameter r. we then follow Karger’s approach to his near-linear time algorithm for
undirected graphs to recover the (exact) directed mincut.

To solve the balanced case, we employ a completely different algorithm this time: we
simply sample vertices s,¢ at random and compute the (s,¢)-mincut. We succeed if we
sample s,t on the correct sides of the mincut, which happens with higher probability when
the mincut is balanced. Finally, we optimize the locality parameter r ~ +/n, achieving
a directed mincut algorithm in O(y/n) max-flow calls. Using the state-of-the-art max-flow
algorithms, we obtain the fastest directed mincut algorithm for both sparse and dense graphs.

5.1 Background

Due to the difficulty of the directed graph setting, only a few of the undirected mincut
algorithms generalize to directed graphs. The most notable one is Hao and Orlin’s O(mn)
time algorithm based on the push-relabel max-flow algorithm. Using a different technique of
duality between rooted mincuts and arborescences, Gabow [40] obtained a running time of

O(mA) for this problem, where A is the weight of a mincut (assuming integer weights). This
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is at least as good as the Hao-Orlin running time for unweighted simple graphs, but can be
much worse for weighted graphs. Indeed, prior to our work, the Hao-Orlin bound of O(mn)
remained the state of the art for the directed mincut problem on arbitrary weighted graphs.

5.2 Our Techniques

At a high level, our algorithm resembles a directed graph version of Karger’s near-linear time
mincut algorithm in undirected graphs [55] as discussed in Section . Recall that Karger’s
algorithm has three main steps: (a) sparsify the graph by random sampling of edges to reduce
the mincut value to O(logn), (b) use a semi-duality between mincuts and spanning trees
to pack O(logn) edge-disjoint spanning trees in the sparsifier, and (c¢) find the minimum
weight cut among those that have only one or two edges in each such spanning tree using a
dynamic program. But, directed graphs are substantially different from undirected graphs.
In particular, steps (a) and (c) are not valid in a directed graph. We cannot hope to sparsify
a directed graph since many directed graphs do not have sparsifiers even in an existential
sense. Moreover, even if a mincut had just a single edge in a spanning tree, Karger’s dynamic
program to recover this cut cannot be used in a directed graph.
To overcome these challenges, we adopt several ingredients that we outline below:

¢ Inspired by locality, we consider two possibilities: either the mincut has O(\/ﬁ) vertices
on the smaller side or fewer (let us call these balanced and unbalanced cuts respectively).
If the mincut is a balanced cut, we use two random samples of O(y/n) and O(1) vertices
each, and find (s,?)-mincuts for all pairs of vertices from the two samples. It is easy
to see that w.h.p., the two samples would respectively hit the smaller and larger sides
of the mincut, and hence, one of these (s,t)-mincuts will reveal the overall mincut of
the graph.

e The main task, then, is to find the mincut when it is unbalanced. In this case, we use
a sequence of steps. The first step is to use cut sparsification of the graph by random
sampling of edges. This scales down the size of the mincut, but unlike in an undirected
graph, all the cuts of a digraph do not necessarily converge to their expected values in
the sample. However, crucially, the mincut can be scaled to O(\/ﬁ) while ensuring that
all the unbalanced cuts converge to their expected values.

¢ Since only the unbalanced cuts converge to their expected values, it is possible that
some balanced cut is the new mincut of the sampled graph, having been scaled down
disproportionately by the random sampling. Our next step is to overlay this sampled
graph with an ezpander graph in the same manner as in Section [6.4f Note that an
expander has a larger weight for balanced cuts than for unbalanced cuts. We choose
the expansion of the graph carefully so that the balanced cuts get sufficiently large
weight of edges that they are no longer candidates for the mincut of the sample, while
the unbalanced cuts are only distorted by a small multiplicative factor.
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e At this point, we have obtained a graph where the original mincut (which was unbal-
anced) is a near-mincut of the new graph. Next, we create a (fractional) packing of
edge-disjoint arborescencesﬂ in this graph using a multiplicative weights update pro-
cedure (e.g., [105]). By duality, these arborescenes have the following property: if we
sample O(logn) random arborescences from this packing, then there will be at least
one arborescence w.h.p. such that the original mincut 1-respects the arborescence. (A
cut 1-respects an arborescence if the latter contains just one edge from the cut.)

e Thus, our task reduces to the following: given an arborescence, find the minimum
weight cut in the original graph among all those that 1-respect the arborescence.
Our final technical contribution is to give an algorithm that solves this problem using
O(log n) maxflow computations. For this purpose, we use a centroid-based recursive
decomposition of the arborescence, where in each step, we use a set of maxflow calls
that can be amortized on the original graph. The minimum cut returned by all these
maxflow calls is eventually returned as the mincut of the graph.

We note that unlike both the Hao-Orlin algorithm and Gabow’s algorithm that are both
deterministic algorithms, our algorithm is randomized (Monte Carlo) and might yield the
wrong answer with a small (inverse polynomial) probability. Derandomizing our algorithm,
or matching our running time bound using a different deterministic algorithm, remains an
interesting open problem.

5.2.1 Additional Preliminaries

The directed mincut problem is formally defined as follows.

Definition 5.2.1: Directed global mincut

Given a directed graph, the global mincut is the smallest-weight set of edges whose
removal causes the graph to no longer be strongly connected.

For simplicity of notation, we define U = V \ U throughout this chapter. Let dU denote
the set of edges in the cut (U, U), so that the directed mincut equals arg mingcpcy w(9U).

Let M F(m,n) denote the time complexity of s-t maximum flow on a digraph with n
vertices and m edges. The current record for this bound is MF(m,n) = O(m + n®/?) [103).
We emphasize that our directed mincut algorithm uses maxflow subroutines in a black box
manner and therefore, any maxflow algorithm suffices. Correspondingly, we express our
running times in terms of M F(m,n).

LAn arborescence is a spanning tree in a directed graph where all the edges are directed away from the
root.
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5.3 The Directed Mincut Algorithm

The main result of this chapter is the following:

Theorem 5.3.1: Directed mincut in O(y/n) max-flows

There is a randomized Monte Carlo algorithm that finds a directed mincut w.h.p. in
O(m+/n) time plus O(y/n) calls to max-flow on an n-vertex, m-edge directed graph.

We now describe the algorithm. Let S* be the source side of a minimum cut. The
algorithm considers the following two cases, computes a cut for each case and takes the
smaller of the two cuts as its final output.

1. The first case aims to compute the correct mincut in the event that min{|S*|, |S*|} > -
v/n/logn. In this case, we randomly sample two vertices s, ¢ € V, then with reasonable
probability, they will lie on opposite sides of the mincut. In that case, we can simply
compute the maxflow from s to t. Repeating the sampling O(y/nlog?n) times, we
obtain the mincut w.h.p. The total running time for this case is O(M F(m, n)/nlog® n)
and is formalized in Lemma below:

If min{|S*|,|S*|} > r, then w.h.p. a mincut can be calculated in time
O(MF(m,n) - (n/r)-logn).

Proof. Uniformly sample a list of & = d - (n/r) - lgn vertices uy, ..., ug, where d is a
large constant. Without loss of generality, assume |S*| < |S*|, and let n = |S—n| > T
With probability at least 1 — 2(1 — )% > 1 —2e7* > 1 — 2n79, the list uy,...,us
contains at least one vertex from each of S* and S*. Hence, there exists i such that
u; and ;1 are on different sides of the (S*,S*) cut. By calculating maxflows for all
(u;, uiy1) and (u;i1, u;) pairs, and reporting the smallest (s,t)-mincut in these calls,
we return a global mincut w.h.p. O]

2. The second case takes care of the event that min{|S*|,|S*|} < 6 -/n/logn. In this
case, we select an arbitrary vertex s, and give an algorithm for finding an s-mincut

defined as:

Definition 5.3.3: s-mincut

An s-mincut is a minimum weight cut among all those that have s on the source
side of the cut, i.e., arg minggycscy w(9S).

Repeating this process with all edge directions reversed, and returning the smaller of
the s-mincuts in the original and the reversed graphs, yields the overall mincut.

We now describe the s-mincut algorithm, where we overload notation to denote the
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value of the s-mincut by . Here, we first guess O(logn) potential values of A, which
is our estimate of A, as the powers of 2, one of which lies in the range [\, 2], and then
for each ), sparsifies the graph using Lemma from Section For each such
sparsifier H, the algorithm then applies Lemmal[5.5.1] from Section 5.5 to pack O(logn)
s-arborescences in H in O(m+/nlogn) time, one of which will 1-respect the s-mincut
in G (for the correct value of \):

Definition 5.3.4: s-arborescence

An s-arborescence is a directed spanning tree rooted at s such that all edges are
directed away from s. A directed s-cut k-respects an s-arborescence if there are
at most k£ cut edges in the arborescence.

Finally, for each of the O(logn) s-arborescences, the algorithm computes the minimum
s-cut that 1-respects each arborescence; this algorithm is described in Algorithm [§| and
proved in Theorem from Section [5.6] It runs in O((MF(m,n) + m) - logn) time
for each of the O(logn) arborescences.

Combining both cases, the total running time becomes O(my/n + MF(m,n)/n), which
establishes Theorem [5.3.1]

5.4 Sparsification

This section aims to reduce mincut value to O(\/ﬁ) while keeping S* a (1 + €)-approximate
mincut for a constant € > 0 that we will fix later. Our algorithm in this stage has two steps.
First, we use random sampling to scale down the expected value of all cuts such that the
expected value of the mincut w(9S*) becomes O(y/n). We also claim that 9.S* remains an
approximate mincut among all unbalanced cuts by using standard concentration inequalities.
However, since the number of balanced cuts far exceeds that of unbalanced cuts, it might
be the case that some balanced cut has now become much smaller in weight than all the
unbalanced cuts. This would violate the requirement that 0S* should be an approximate
mincut in this new graph. This is where we need our second step, where we overlay an
expander on the sampled graph to raise the values of all balanced cuts above the expected
value of 05* while only increasing the value of 9S* by a small factor. This last technique is
inspired by recent work of Li [69] for a deterministic mincut algorithm in undirected graphs.

Now, we prove the main property of this section:
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Lemma 5.4.1

Given a digraph GG, a parameter Ae [\, 2)], and a constant € € (0, 1), we can construct
in O(mlogn) time a value p € (0,1] and a digraph H with O(m) edges such that the
following holds w.h.p. for the value p = min{‘/Tﬁ, 1}.

1. There is a constant § > 0 (depending on €) such that for any set ) # S C V
with min{|S|,|S|} < 6 - +/n/logn, we have

(1 =€) -p-da(5) < 0u(S) < (1+¢€)-p-a(S);

2. For any set 0 # S C V, 0u(S) > (1 — €)pA.

Proof. 1f A< 2y/n, then \ < 2 < 2y/n as well, so we set H to be G itseilf, which satisfies
all the properties for p = 1. For the rest of the proof, we assume that A\ > 2/n, so that
A > \/n, and we set p = \/TE < 1. Throughout the proof, define ¢ = ¢/2, r = %ﬁ/ logn,

o= &C? and 0 = E;ZO, where «q is the constant from Lemma [8.2.1]

We first construct digraph G by reweighting the edges of GG as follows. For each edge e in
G, assign it a random new weight w(e) chosen according to binomial distribution B(w(e), p).
(If wg(e) = 0, then remove e from G.) For each set § # S C V with min{|S|,|S|} < 7,
we have Ed(S5) = pdi(S), and by Chernoff bound, the probability that d4(.S) falls outside
[(1 — €)pde(S), (1+ €¢)pda(S)] is upper-bounded by 2e*<*/3 < 2n=2". There are O(n") sets
S with min{|S[,|S|} < r, so by a union bound, w.h.p. all such sets satisfy (1 — €)pda(S) <
0a(S) < (14 €)pig(S).

Construct graph X according to Lemma and split each undirected edge into two
directed edges. Let H be the “union” of G and aX, so that each edge e in H has weight
wr(e) = wy(e) + awx(e), where we say w(e) = 0 if e does not exist in the corresponding
graph.

We now show that H satisfies the two desired properties.

1. For any set § # S C V with min{|S|,|S|} < 8- /n/logn =
0u(S) > 64(S) > (1 — €)pde(S) from before, so Iy (S) > (1 — e)
the upper bound, we have

or < r, we have
( ) as well. For

6 (S) = 64(S)+adx(S) < (1+€)pda(S)+9a|S| < (14€)pda(S)+€vn < (14€)pda(S)

2. For any set ) # S C V with min{|S|,|S|} < 6-+/n/logn = egor < r, we have

u(S) > 04(S) > (1 — €)pda(S) > (1 — €)pA as required by property (2). When
min{|S],|S|} > r, we have §5(S) > adx(S) > aagr > y/n forall ) #S C V.

Finally, H has O(m) edges because E(G) is a subset of E(G) and E(X) = O(n). O
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5.5 Finding a 1-respecting Arborescence

In this section, we assume that there is an unbalanced mincut and show how to obtain an
s-arborescence that 1-respects the mincut. More formally, we prove the following:

Lemma 5.5.1

Given weighted digraph G and a fixed vertex s such that s is in the source side of a min-
imum cut S* and min{|S*|, [S*|} < 0-/n/logn where 0 is defined in Lemma in
O(m+/nlogn) time we can find O(logn) s-arborescences, such that w.h.p. a minimum
cut 1-respects one of them.

The idea of this lemma is as follows. First, we apply Lemma to our graph G and
obtain graph H. w.h.p., a mincut S* in G corresponds to a cut in H of size (1 & ¢)pA and
no cut in H has size less than (1 — €)pA. That is, S* is a (1 + O(¢))-approximate mincut
in H. It remains to find an arborescence in H that 1-respects S*. To do this, we employ a
multiplicative weight update (MWU) framework. The algorithm begins by setting all edge
weights to be uniform (say, weight 1). Then, we repeat for O(y/nlog(n)/e?) rounds. For
each round, we find in near-linear time a minimum weight arborescence and multiplicatively
increase the weight of every edge in the arborescence.

Using the fact that there is no duality gap between arborescence packing and mincut
[33, 140], a standard MWU analysis implies that these arborescences that we found, after
some scaling, form a (1 + €)-approximately optimal fractional arborescence packing. So
our arborescence crosses S* at most (1 + O(¢€)) < 2 times on average. Thus, if we sample
O(logn) arborescences from our collections, w.h.p., one of them will 1-respect S *El Below,
we formalize this high level description.

Definition 5.5.2: Packing problem [105]

For convex set P C R"™ and nonnegative linear function f : P — R™, let v*
minge p Max;epm, f;j(x) be the solution in P that minimizes the maximum value of f;(x
over all j, and define the width of the packing problem as w = max;cjm)zcp fj(®)

minje[m],zeP fj (ZL’) .

~—

The fractional arborescence packing problem conforms to this definition. Enumerate all
the s-arborescences as Aj, Ag, ..., Ay. We represent a fractional packing of arborescences
as a vector in RY, where coordinate i represents the fractional contribution of A; in the
packing. LetP = {x € R" : 271 = 1,2 > 0} be the convex hull of all single arborescences.
For each edge j with capacity w(j), fj(z) = > ;v 2illd € Til/w(j) is the relative load of
arborescence packing x on edge j. It is easy to see that w < 1/wy, for tree packing. The
objective function is to minimize the maximum load: v* = min,ecp max;cp, f;(z).

2This should be compared with Karger’s mincut algorithm in the undirected case, where there is a factor
2 gap, and hence Karger can only guarantee a 2-respecting tree in the undirected case.
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For any fractional arborescence packing » € RY with value 271 = v where f;(z) <1 for
all edges 7, we have %x € P. In particular, the maximum arborescence packing, once scaled
down by its value, is exactly the vector in P that minimizes the maximum load. Therefore,
it suffices to look for the vector x € P achieving the optimal value 7v*, and then scale the
vector up by 1/4* to obtain the maximum arborescence packing.

Next we describe the packing algorithm (Figure 2 of [I05]). Maintain a vector y € R™,
initially set to y = 1. In each iteration, find x = argmin,ep ) ; y; f;(z), and then add = to
set S and replace y by the vector 3’ defined by yi = y;(1 + efj(z)/w). After a number of
iterations, return r € P, the average of all the vectors x over the course of the algorithm.
The lemma below upper bounds the number of iterations that suffice:

Lemma 5.5.3: Corollary 6.3 of [105]

After [ ((ﬁti)l;’(lfﬁ)_eﬂ iterations of the packing algorithm, 5 = max; f;(zZ) < (14+€)v*.

We will also make use of the (exact) duality between s-arborescence packing and minimum
s-cut:

Lemma 5.5.4: Corollary 2.1 of [40]

The value of maximum s-arborescence packing is equal to the value of minimum s-cut.

Proof of Lemma[5.5.1 First, construct H according to Theorem[5.4.1] By the duality above,
the minimum s-cut on H has value Ay = 7% Since min{|S*|,|S*|} < 6/n/logn, we have

A < 0p(S*) < (1+¢)pA < (1+¢€)y/n.

Run the aforementioned arborescence packing algorithm up to O(Aylnm) iterations,
after which Lemma [5.5.3] guarantees that 4 < (1 + €)y*. Then Z/7 is a vector in P with
value 1/7 > %JFEAH.

Consider sampling a random arborescence A from the distribution specified by z/7, so
we choose arborescence A; with probability z; /4. Since 65 (S*) < (1+¢€)pA < (1+4€)?My, the
expected number of edges in ANdy(S*) is at most % < 14-4e for small enough € > 0. Since
we always have |ANdg (S*)| > 1, by Markov’s inequality Pr[|ANdy(S*)|—1> 1] <4e < 1/2
for small enough e. Therefore, if we uniformly sampling O(logn) arborescences from the

distribution z /7, at least one of the arborescences is 1-respecting w.h.p.

It remains to compute ¥ = argmingep Y _; y;fj(z) on each iteration. Since >, y;fj(z) is
linear in z, the minimum must be achieved by a single arborescence. So the task reduces to
computing the minimum cost spanning s-arborescence, which can be done in O(m+nlogn)
[39]. The total time complexity, over all iterations, becomes O((m + nlogn)Aglogn) =
O((m + nlogn)y/nlogn). O
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5.6 Mincut Given 1-respecting Arborescence

We propose an algorithm (Algorithm [§)) that uses O(logn) maxflow subroutines to find the
minimum s-cut that 1-respects a given s-arborescence. The result is formally stated in

Theorem B.6.1]

Theorem 5.6.1

Consider a directed graph G = (V, E, w) with n vertices, m edges, and polynomially
bounded edge weights w, > 0. Fix a global (directed) mincut S of G. Given an
arborescence T rooted at s € S with [T N (S, S)| = 1, Algorithm [§ outputs a global
minimum cut of G in time O((MF(m,n) +m) -logn).

We first give some intuition for Algorithm [§ Because s € S, if we could find a vertex
t € S, then computing the s-t mincut using one maxflow call would yield a global mincut of
G. However, we cannot afford to run one maxflow between s and every other vertex in G.
Instead, we carefully partition the vertices into ¢ = O(logn) sets (C;)e_,. We show that for
each C;, we can modify the graph appropriately so that it allows us to (roughly speaking)
compute the maximum flow between s and every vertex ¢ € C; using one maxflow call.

More specifically, Algorithm [§ has two stages. In the first stage, we compute a centroid
decomposition of T. Recall that a centroid of T is a vertex whose removal disconnects T
into subtrees with at most n/2 vertices. This process is done recursively, starting with the
root s of T'. We let P, denote the subtrees resulting from the removal of s from 7'. In each
subsequent step i, we compute the set C; of the centroids of the subtrees in P;. We then
remove the centroids and add the resulting subtrees to P; ;. This process continues until no
vertices remain.

In the second stage, for each layer i, we construct a directed graph G; and perform one
maxflow computation on GG;. The maxflow computation on GG; would yield candidate cuts for
every vertex in Cj;, and after computing the appropriate maximum flow across every layer,
we output the minimum candidate cut as the minimum cut of G. The details are presented
in Algorithm [§

We first state two technical lemmas that we will use to prove Theorem [5.6.1

Recall that P; is the set of subtrees in layer ¢ and C; contains the centroid of each
subtree in P;. If C; C S for every 0 < j < 4, then S is contained in exactly one subtree
in P;, and consequently, at most one vertex u € C; can be in S.
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Algorithm 8 Finding the global minimum directed cut.

Input: An arborescence T rooted at s € S such that S 1-respects T

—_

— = =
N 22

13:
14:
15:

16:

. // Stage I: Build centroid decomposition.
Let Cy = {s}, Pi = the set of subtrees obtained by removing s from 7', and i = 1.
while P, # @ do

Initialize C; (the centroids of P;) and P;,; as empty sets.

for each subtree U € P, do Compute the centroid u of U and add it to C;.

Add all subtrees generated by removing u from U to P, ;.

Set ¢ =i and iterate 1 =i + 1.

// Stage II: Calculate integrated maximum flow for each layer.
for:=1to ¢ do

Construct a digraph G; = (V U {t;}, E1 U E, U E3) as follows (see Figure [5.1):

1) Add edges Ey = ENUyep, (U x U) with capacity equal to their original weight.

2) Add edges Fy = {(s,v) : (u,v) € E'\ E;} with capacity of (s,v) equal to the
original weight of (u,v).

3) Add edges E3 = {(u,t;) : u € C;} with infinite capacity.

Compute the maximum s-¢; flow f; in Gj.

For each component U € P, with centroid w, the value of f on edge (u,t;) is a
candidate cut value, and the nodes in U that can reach u in the residue graph is a
candidate for S.

Return the smallest candidate cut as minimum s-cut and the corresponding (S, S).

Figure 5.1:  Construction of auxiliary graph G; in Algorithm [§ Solid lines represent the
arborescence T'. Dashed lines are other edges in the graph. Rectangles are sets formed by
the first level of centroid decomposition. Left: The original graph. Right: The part of G,
solving the case that the mincut separates root and the centroid of the middle subtree.
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Let G; be the graph constructed in Step [10] of Algorithm [ Let f;* be a maximum s-;
flow on G; as in Step [I4} For any U € P; with centroid u, the amount of flow f; puts
on edge (u,t;) is equal to the value of the minimum cut between U and u.

We defer the proofs of Lemmas[5.6.2/and [5.6.3], and first use them to prove Theorem[5.6.1}
Proof of Theorem[5.6.1 We first prove the correctness of Algorithm

Because Cp = {s} and s € S, and the C;’s form a disjoint partition of V| there must
be a layer i such that for the first time, we have a centroid u € C; that belongs to S. By
Lemma we know that S must be contained in exactly one subtree U € P;, and hence
u must be the centroid of U. In summary, we have v € S and S C U.

Consider the graph G; constructed for layer ¢. By Lemma based on the flow f7
puts on the edge (u,t;), we can recover the value of the minimum (directed) cut between U
and u. Because S C U (or equivalently U C S) and u € S, the cut (5, S) is one possible cut
that separates U and u. Therefore, the flow that f; puts on the edge (u,t;) is equal to the
global mincut value in G.

In addition, the candidate cut value for any other centroid u’ of a subtree U’ € P; must
be at least the mincut value between s and u’. This is because the additional restriction that

the cut has to separate U’ from «' can only make the mincut value larger, and the value of
this cut in G| is equal to the value of the same cut in G. Therefore, the minimum candidate
cut value in all £ layers must be equal to the global mincut value of G.

Now we analyze the running time of Algorithm We can find the centroid of an n-
node tree in time O(n) (see e.g., [81]). The total number of layers ¢ = O(logn) because
removing the centroids reduces the size of the subtrees by at least a factor of 2. Thus, the
running time of Stage I of Algorithm [§is O(nlogn). In Stage II, we can construct each
G; in O(m) time and every G; has O(m) edges. Since there are O(logn) layers and the
maximum flow computations take a total of O(M F(m,n) - logn) time, the overall runtime
is O(nlogn + (M F(m,n) +m)logn) = O((MF(m,n) +m)logn). O

Before proving Lemmas [5.6.2 and we first prove the following lemma.

If # and y are vertices in S, then every vertex on the (undirected) path from z to y in
the arborescence T' also belongs to S.

Proof. Consider the lowest common ancestor z of x and y. Because there is a directed path
from z to z and a directed path from z to y, we must have z € S. Otherwise, there are at
least two edges in T' that go from S to S.

Because s € S and z € S, there is already an edge in 7' (on the path from s to z) that
goes from S to S. Consequently, all other edges in T’ cannot go from S to S, which means
the entire path from z to z (and similarly z to y) must be in S. ]
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Recall that Lemma [5.6.2] states that if all the centroids in previous layers are in S, then
S is contained in exactly one subtree U in the current layer i.

Proof of Lemma[5.6.4 For contradiction, suppose that there exist distinct subtrees U; and
U, in P; and vertices z,y € S such that = € U; and y € Us.

By Lemma , any vertex on the (undirected) path from x to y also belongs to S.
Consider the first time that x and y are separated into different subtrees. This must have
happened because some vertex on the path from x to y is removed. However, the set of
vertices removed at this point of the algorithm is precisely Uog i< C;, but our hypothesis
assumes that none of them are in S. This leads to a contradiction and therefore S is contained
in exactly one subtree of P;.

It follows immediately that at most one centroid u € C; can be in S. O

Next we prove Lemma [5.6.3] which states that the maximum flow between s and ¢; in

the modified graph G; allows one to simultaneously compute a candidate mincut value for
each vertex u € Cj.
Proof of Lemma[5.6.3. First observe that the maxflow computation from s to ¢; in G; can
be viewed as multiple independent maxflow computations. The reason is that, for any two
subtrees Uy, Uy € P;, there are only edges that go from s into U; and from U; to t; in G,
(similarly for Us), but there are no edges that go between U; and Us.

The above observation allows us to focus on one subtree U € P;. Consider the procedure
that we produce G; from G in Steps [I1] to [13] of Algorithm [8] The edges with both ends in
U are intact (the edge set Fy). If we contract all vertices out of U into s, then all edges that
enter U would start from s, which is precisely the effect of removing cross-subtree edges and
adding the edges in F5. One final infinity-capacity edge (u,t;) € E3 connects the centroid of
U to the super sink t;.

Therefore, the maximum s-t; flow f; computes the maximum flow between U and u € U
simultaneously for all U € P;, whose value is reflected on the edge (u,t;). It follows from the
maxflow mincut theorem that the flow on edge (u,t;) is equal to the mincut value between U
and v in G (i.e., the minimum value w(A, A) among all A C V with U C Aand u € A). O

5.7 Conclusion

In this section, we presented our directed mincut algorithm which runs in roughly O(y/n)
many max-flows. There is still a lot of progress to be made, and we expect polylogarithmic
many max-flows to be eventually within reach. Improving upon our result would likely
require a more sophisticated sparsification procedure which may involve dependent sampling
among edges.

Another interesting direction to take is trying to avoid max-flow computations altogether,
which is possible for undirected global mincut. This would likely require a fundamentally
different approach, however, as even the sparsification and arborescence packing part of our
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algorithm—which were directly inspired by Karger’s in the undirected case—could not avoid
max-flow computations.
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Chapter 6

Deterministic Mincut

This chapter is dedicated to the global mincut problem: given an undirected graph, find
the minimum-weight set of edges whose removal disconnects the graph. We study global
mincut from a preconditioning point of view and present the first almost-linear deterministic
algorithm for this problem, following the work of [69].

Prior to the works studied in this thesis, the global mincut problem was one of the most
notorious to derandomize in the context of graph algorithms. Even though a near-linear time
randomized algorithm was known since the 1990s [55], the best deterministic running time
for general graphs remained O(mn) Karger’s algorithm was difficult to derandomize due
to its sparsification step, where we sparsify the graph to approximately preserve the global
mincut.

In this chapter, we manage to derandomize Karger’s graph sparsification routine through
a preconditioning approach: we first restrict our attention to the expander graphs, and then
apply expander decompositions to handle the general case. To convey the conceptual insights
of this chapter in a relatively simple setting, we devote a separate section, Section [6.4] for
just the expander case. There, we discuss a key property of expanders that we exploit in
our algorithm: the global mincut in an expander must be unbalanced; in other words, we
can employ a locality-based algorithm to solve this case. This shows that the concepts of
preconditioning and locality often go hand-in-hand. Finally, we handle the general case by
computing a recursive expander decomposition of the graph, which is covered in Section
and constitutes the technical bulk of the chapter.

6.1 Background

The global mincut problem dates back to the work of Gomory and Hu [44] in 1961 who gave
an algorithm to compute the mincut of an n-vertex graph using n— 1 max-flow computations.
Since then, a large body of research has been devoted to obtaining faster algorithms for
this problem. In 1992, Hao and Orlin [48] gave a clever amortization of the n — 1 max-
flow computations to match the running time of a single max-flow computation, though
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their method is specific to the push-relabel max-flow algorithm [42] and therefore takes
O(mnlog(n®/m)) time. Around the same time, Nagamochi and Ibaraki [86] (see also [85])
designed an algorithm that bypasses max-flow computations altogether, a technique that
was further refined by Stoer and Wagner [102] (and independently by Frank in unpublished
work). This alternative method yields a running time of O(mn-+n?logn). Before the results
of this thesis, these O(mn) time algorithms were the fastest deterministic mincut algorithms
for weighted graphs.

Starting with Karger’s contraction algorithm in 1993 [54], a parallel body of work started
to emerge in randomized algorithms for the mincut problem. This line of work (see also
Karger and Stein [56]) eventually culminated in a breakthrough paper by Karger [55] in
1996 that gave an O(mlog®n) time Monte Carlo algorithm for the mincut problem. Note
that this algorithm comes to within poly-logarithmic factors of the optimal O(m) running
time for this problem. In that paper, Karger asks whether we can also achieve near-linear
running time using a deterministic algorithm, which we answer affirmatively in this chapter.

Karger’s question has also been resolved for specific instances in the past. In a recent
breakthrough, Kawarabayashi and Thorup [57| gave the first near-linear time deterministic
algorithm for this problem for simple graphs. They obtained a running time of O(mlog™ n),
which was later improved by Henzinger, Rao, and Wang [51] to O(mlog®nloglog?n), and
then simplified by Saranurak [93] at the cost of m!'*°() running time. From a technical
perspective, Kawarabayashi and Thorup’s work introduced the idea of using low conductance
cuts to find the mincut of the graph, the main inspiration behind the preconditioning-based
approach we present in this chapter.

6.2 Owur Techniques

Our main result is formally stated as follows.

Theorem 6.2.1: Deterministic mincut

There is a deterministic algorithm that computes the mincut of a weighted, undirected
graph in 90 (logn)*/C(loglog M)y 45171

At a high level, we follow Karger’s approach and essentially de-randomize the single
randomized procedure in Karger’s near-linear time mincut algorithm [55], namely the con-
struction of the skeleton graph, which Karger accomplishes through the Benczur-Karger
graph sparsification technique by random sampling. We remark that our de-randomization
does not recover a full (1 + €)-approximate graph sparsifier, but the skeleton graph that we
obtain is sufficient to solve the mincut problem.

Let us first briefly review the Benczur-Karger graph sparsification technique, and discuss
the difficulties one encounters when trying to de-randomize it. Given a weighted, undirected
graph, the sparsification algorithm samples each edge independently with a probability de-
pending on the weight of the edge and the global mincut of the graph, and then re-weights
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the sampled edge accordingly. In traditional graph sparsification, we require that every cut
in the graph has its weight preserved up to a (1 + €) factor. There are exponentially many
cuts in a graph, so a naive union bound over all cuts does not work. Benczur and Karger’s
main insight is to set up a more refined union bound, layering the (exponentially many) cuts
in a graph by their weight. They show that for all a > 1, there are only n° many cuts in a
graph whose weight is roughly « times the mincut, and each one is preserved up to a (1 +¢)
factor with probability 1 — n=¢®, for some constants ¢ > ¢. In other words, they establish
a union bound layered by the a-approximate mincuts of a graph, for each a > 1.

One popular method to de-randomize random sampling algorithms is through pessimistic
estimators, which is a generalization of the well-known method of conditional probabilities.
For the graph sparsification problem, the method of pessimistic estimators can be imple-
mented as follows. The algorithm considers each edge one by one in some arbitrary order,
and decides on the spot whether to keep or discard each edge for the sparsifier. To make
this decision, the algorithm maintains a pessimistic estimator, which is a real number in the
range [0, 1) that represents an upper bound on the probability of failure should the remaining
undecided edges each be sampled independently at random. In many cases, the pessimistic
estimator is exactly the probability upper bound that one derives from analyzing the random
sampling algorithm, except conditioned on the edges kept and discarded so far. The algo-
rithm makes the choice—whether to keep or discard the current edge—based on whichever
outcome does not increase the pessimistic estimator; such a choice must always exist for the
pessimistic estimator to be valid. Once all edges are processed, the pessimistic estimator
must still be a real number less than 1. But now, since there are no more undecided edges,
the probability of failure is either 0 or 1. Since the pessimistic estimator is an upper bound
which is less than 1, the probability of failure must be 0; in other words, the set of chosen
edges is indeed a sparsifier of the graph.

In order for this de-randomization procedure to be efficient, the pessimistic estimator
must be quickly evaluated and updated after considering each edge. Unfortunately, the
probability union bound in the Benczur-Karger analysis involves all cuts in the graph, and
is therefore an expression of exponential size and too expensive to serve as our pessimistic
estimator. To design a more efficient pessimistic estimator, we need a more compact, easy-
to-compute union bound over all cuts of the graph. We accomplish this by grouping all cuts
of the graph into two types: small cuts and large cuts.

Small cuts. Recall that our goal is to preserve cuts in the graph up to a (1 + ¢€) factor.
Let us first restrict ourselves to all a-approximate mincuts of the graph for some a = n°™.
There can be n%(®) many such cuts, so the naive union bound is still too slow. Here, our main
strategy is to establish a structural representation of all a-approximate mincuts of a graph,
with the goal of deriving a more compact “union bound” over all a-approximate cuts. For an
expander, this task is relatively easy: in an expander with conductance ¢, all a-approximate
mincuts must have at most «/¢ vertices on one side, so a compact representation is simply
all cuts with at most «/¢ vertices on one side. Motivated by this connection, we show that if
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the original graph is itself an expander, then it is enough to preserve all vertex degrees and all
edge weights up to an additive € \ factor, where A is the mincut of the graph and ¢’ depends
on €,a, . We present the unweighted expander case in Section as a warm-up, which
features all of our ideas except for the final expander decomposition step. To handle general
graphs, we compute an expander hierarchy of the graph, which is a recursive, hierarchical
expander decomposition structure introduced by Goranci et al. [45].

Large cuts. For the large cuts—those that are not a-approximate mincuts—our strategy
differs from the pessimistic estimator approach. Here, our aim is not to preserve each of them
up to a (1 + ¢)-factor, but a ~-factor for a different parameter v = n°!). This relaxation
prevents us from obtaining a full (1 4 €)-approximate sparsification of the graph, but it still
works for the mincut problem since the large cuts do not fall below the original mincut
value. While a deterministic (1 + €)-approximate sparsification algorithm in near-linear time
is unknown, one exists for y-approximation sparsification for some v = n°®) [27]. In our case,
we actually need the sparsifier to be uniformly weighted, so we construct our own sparsifier
in Section [6.5.3, again via the expander hierarchy. Note that if the original graph is an
expander, then we can take any expander whose degrees are roughly the same; in particular,
the sparsifier does not need to be a subgraph of the original graph. To summarize, for the
large cuts case, we simply construct an y-approximate sparsifier deterministically, bypassing
the need to de-randomize the Benczur-Karger random sampling technique.

Combining them together. Of course, this y-approximate sparsifier destroys the guar-
antee of the small cuts, which need to be preserved (1 + ¢)-approximately. Our strategy
is to combine the small cut sparsifier and the large cut sparsifier together in the follow-
ing way. We take the union of the small cut sparsifier with a “lightly” weighted version of
the large cut sparsifier, where each edge in it is weighted by €/ times its normal weight.
This way, each small cut of weight w suffers at most an additive yw - €/7 = ew weight
from the “light” large cut sparsifier, so we do not destroy the small cuts guarantee (up to
replacing € with 2¢). Moreover, each large cut of weight w > a\ is weighted by at least
w/y-€e/y > a\/y-e/y = a/y? - e\, where ) is the mincut of the original graph. Hence, as
long as a > % /¢, the large cuts have weight at least the mincut, and the property for large
cuts is preserved.

Unbalanced vs. balanced. We remark that our actual separation between small cuts
and large cuts is somewhat different; we use unbalanced and balanced instead to emphasize
this distinction. Nevertheless, we should intuitively think of unbalanced cuts as having
small weight and balanced as having large weight; rather, the line is not drawn precisely
at a weight threshold of a\. The actual separation is more technical, so we omit it in this
overview section.
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6.3 Additional Preliminaries

In this chapter, sometimes we will mention weighted graphs and unweighted graphs in the
same context. We make this distinction because sometimes, it is necessary to consider un-
weighted graphs on their own, rather than as a special case of weighted graphs. In particular,
the skeleton graph that we compute in the algorithm must be unweighted. For this reason,
we introduce separate graph-theoretic notation for unweighted graphs to better differentiate
them from their weighted counterparts.

For an unweighted graph G = (V, E), let #(u, v) be the number of (parallel) edges e € F
with endpoints u and v. For a set F' C E of edges, denote its cardinality by |F|, and for a
vertex v € V, define its degree deg(v) to be |0({v})]. The rest of the definitions remain the
same for weighted and unweighted graphs.

6.3.1 Karger’s Approach

In this section, we outline Karger’s approach to his near-linear time randomized mincut
algorithm and set up the necessary theorems for our deterministic result. Karger’s algorithm
has two main steps. First, it computes a small set of (unweighted) trees on vertex set V'
such that the mincut 2-respects one of the trees T', defined as follows:

Definition 6.3.1

Given a weighted graph G and an unweighted tree 7" on the same set of vertices, a cut
JaS 2-respects the tree T if |0rS| < 2.

Karger accomplishes this goal by first sparsifying the graph into an unweighted skeleton
graph using the well-known Benzcur-Karger sparsification by random sampling, and then
running a tree packing algorithm of Gabow [40] on the skeleton graph.

Theorem 6.3.2: Global mincut given skeleton graph [55]

Let G be a weighted graph, let m’ and ¢’ be parameters, and let H be an unweighted
graph on the same vertices, called the skeleton graph, with the following properties:
(a) H has m' edges,
(b) The mincut of H is ¢, and

(c) The mincut in G corresponds (under the same vertex partition) to a 7/e-
approximate mincut in H.
Given graphs G and H, there is a deterministic algorithm in O(¢'m’logn) time that

constructs O(c’) trees on the same vertices such that one of them 2-respects the mincut
in G.

The second main step of Karger’s algorithm is to compute the mincut of G given a tree
that 2-respects the mincut. This step is deterministic and is based on dynamic programming.
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Theorem 6.3.3: Minimum 2-respecting cut algorithm [55]

Given a weighted, undirected graph G and a (not necessarily spanning) tree 7" on the
same vertices, there is a deterministic algorithm in O(mlog®n) time that computes
the minimum-weight cut in G that 2-respects the tree T

Our main technical contribution is a deterministic construction of the skeleton graph
used in Theorem [6.3.2] Instead of designing an algorithm to produce the skeleton graph
directly, it is more convenient to prove the following, which implies a skeleton graph by the
following claim.

Theorem 6.3.4: Mincut sparsifier

For any 0 < € < 1, we can compute, in deterministic ¢—420(logn)*/®(loglog ) °) 1 time,
an unweighted graph H and some weight W = ¢*) /20(og n)*/0(loglog )™ gych that

1. For any mincut 0S* of G, we have W - |0y S*| < (1 + €)A, and
2. For any cut ) € S C V of G, we have W - |0y S| > (1 — e)\.

Claim 6.3.5

For ¢ = 0.01, the graph H in Theorem [6.3.4] fulfills the conditions of Theorem [6.3.2]

with m/ = m!*t°® and ¢ = n°W,

Proof. Since the algorithm of Theorem m takes m!'*°() time, the output graph H must
have m'*°(1) edges, fulfilling condition (a) of Theorem [6.3.2, For any mincut S* of G, by
property (1) of Theorem , we have |0y S*| < (14+€)A/W < n°M fulfilling condition (b).
For any cut ) C S C V, by property (2), we have |0yS| > (1 — e)A\/W. In other words,
S*isa (1+€)/(1 — e)-approximate mincut, which is a 7/6-approximate mincut for € = 0.01,
fulfilling condition (c). O

With the above three statements in hand, we now prove Theorem [6.2.1] following Karger’s
approach. Run the algorithm of Theorem [6.3.4] to produce a graph H which, by Claim [6.3.5]
satisfies the conditions of Theorem Apply Theorem on GG and the skeleton graph
H, producing n°® many trees such that one of them 2-respects the mincut in G. Finally,
run Theorem [6.3.3| on each tree separately and output the minimum 2-respecting cut found
among all the trees, which must be the mincut in G. Each step requires 2002 n)*/0 (loglog n) *)
deterministic time, proving Theorem [6.2.1

Thus, the main focus for the rest of the chapter is proving Theorem [6.3.4]

6.3.2 Spectral Graph Theory

Central to our approach are the well-known concepts of conductance, expanders, and the
graph Laplacian from spectral graph theory.
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Definition 6.3.6: Conductance, expander

The conductance of a weighted graph G is

._ w(E(S,V\ 5))
2(G):= pCSeV min{vol(S),vol(V \ 9)}

For the conductance of an unweighted graph, replace w(E(S,V '\ S)) by |E(S,V \ 5)|.
We say that G is a ¢-ezpander if ®(G) > ¢.

Definition 6.3.7: Laplacian

The Laplacian L¢ of a weighted graph G = (V, E) is the n x n matrix, indexed by
V x V', where
(a) Each diagonal entry (v,v) has entry deg(v), and
(b) Each off-diagonal entry (u,v) (u # v) has weight —w(u,v) if (u,v) € E and 0
otherwise.

The only fact we will use about Laplacians is the following well-known fact, that cuts in
graphs have the following nice form:

Fact 6.3.8

For any weighted graph G = (V, E') with Laplacian Lg, and for any subset S C V| we
have
w(é?S) = ]15[4@15,

where 1g € {0,1}V is the vector with value 1 at vertex v if v € S, and value 0
otherwise. For unweighted graph G, replace w(9S) with |05].

6.4 Expander Case

In this section, we prove Theorem [6.3.4] restricted to the case when G is an unweighted
expander. Our aim is to present an informal, intuitive exposition that highlights our main
ideas in a relatively simple setting. Since this section is not technically required for the main
result, we do not attempt to formalize our arguments, deferring the rigorous proofs to the
general case in Section [6.5]
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Theorem 6.4.1: Deterministic mincut on expanders

Let G be an unweighted ¢-expander multigraph. For any 0 < € < 1, we can compute,
in deterministic m!T°) time, an unweighted graph H and some weight W = €3\ /n°()
such that

(a) For any mincut 95S* of G, we have W - [0 S*| < (1 + €)A, and

(b) For any cut 0gS of G, we have W - |0y S| > (1 — €)A.

For the rest of this section, we prove Theorem [6.4.1]
Consider an arbitrary cut dgS. By Fact [6.3.8] we have

0S| = 15 Lals = (Z 15) L¢ (Z nv> = > 1/LeL,. (6.1)

veS vES u,vES

Suppose we can approximate each 17Ls1, to an additive error of €\ for some small ¢
(depending on €); that is, suppose that our graph H and weight W satisfy

171, — W - 1T L1, < €N

for all u,v € V. Then, by , we can approximate |0gS| up to an additive |S|%¢'\, or
a multiplicative (1 + |S|?¢’), which is good if |S| is small. Similarly, if |V \ S| is small,
then we can replace S with V' '\ S in and approximate |0gS| = |0g(V \ 9)| to the
same factor. Motivated by this observation, we define a set S C V to be unbalanced if
min{vol(S),vol(V \ S)} < a)\/¢ for some a = n°Y) to be set later. Similarly, define a
cut 0gS to be unbalanced if the set S is unbalanced. Note that an unbalanced set S must
have either |S| < a/¢ or |V \ S| < a/¢, since if we assume without loss of generality that
vol(S) < vol(V'\ S), then

[SIA <) deg(v) = vol(S) < a)/¢, (6.2)

veES

where the first inequality uses that each degree cut d({v}) has weight deg(v) > A. Moreover,
since (G is a ¢-expander, the mincut dgS* is unbalanced because, assuming without loss of
generality that vol(S*) < vol(V \ S*), we obtain

Leldl 2 0(6) 2 0 — vol(5) < 1/ < a/o

To approximate all unbalanced cuts, it suffices by and to approximate each
1TLa1, up to additive error (¢/a)?eA. When u # v, the expression 11 Lg1, is simply
the negative of the number of parallel (u,v) edges in G. So, approximating 17 Ls1, up
to additive error e\ simply amounts to approximating the number of parallel (u,v) edges.
When u = v, the expression 17 Ls1, is simply the degree of v, so approximating it amounts
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to approximating the degree of v.
alogn
2¢)\ )

and weight the sampled edges by W=1 /p to form the sampled graph H. For the terms
17La1, (u # v), we have #¢(u,v) < vol(S) < a)/¢. Let us assume for simplicity that
#a(u,v) = a)/¢, which turns out to be the worst case. By Chernoff bounds, for § = €¢/a,

Consider what happens if we randomly sample each edge with probability p = O(=35">

Pr H#ﬁ(u,v) —p- #G(u,v)| >0 -p-H#a(u,v)] <2exp(=0°-p- #c(u,v)/3)

2
— 2exp (— (%) 0 (O‘;Oé”) . ag/gf’)

(6.3)

— 2exp(—O(logn)),
which we can set to be much less than 1/n%. We then have the implication

[#7(u,v) —p-#a(u,v)| <6 p-#alu,v) = |11 (Le — Ly)Lu| <6 - #a(u,v)
=ep/a-al/p = e

Similarly, for the terms 17 L1, we have deg(v) < vol(S) < a)/¢, and the same calculation
can be made.

From this random sampling analysis, we can derive the following pessimistic estimator.
Initially, it is the sum of the quantities for all (u, v) satisfying either u = v or (u,v) € E.
This sum has O(m) terms which sum to less than 1, so it can be efficiently computed
and satisfies the initial condition of a pessimistic estimator. After some edges have been
considered, the probability upper bounds are modified to be conditional to the choices
of edges so far, which can still be efficiently computed. At the end, for each unbalanced set
S, the graph H will satisfy

106:S| = W - [055]] < A = (1—€)|0eS| <W - |055] < (1 + €)[0eS].

Since any mincut d¢S* is unbalanced, we fulfill condition (a) of Theorem [6.4.1 We also fulfill
condition (b) for any cut with a side that is unbalanced. This concludes the unbalanced
case; we omit the rest of the details, deferring the pessimistic estimator and its efficient
computation to the general case, specifically Section [6.5.2]

Define a cut to be balanced if it is not unbalanced. For the balanced cuts, it remains to
fulfill condition (b), which may not hold for the graph H. Our solution is to “overlay” a fixed
expander onto the graph H , weighted small enough to barely affect the mincut (in order to
preserve condition (a)), but large enough to force all balanced cuts to have weight at least A.
In particular, let H be an unweighted ©(1)-expander on the same vertex set V' where each
vertex v € V has degree O(degq,(v)/A), and let W = O(ep)). We should think of H as a
“lossy” sparsifier of G, in that it approximates cuts up to factor O(1/¢), not (1 + ¢).

Consider taking the “union” of the graph H weighted by W and the graph H weighted
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by W. More formally, consider a weighted graph H’ where each edge (u,v) is weighted by
w. wg(u,v) + W w 7(u,v). We now show two properties: (1) the mincut gains relatively
little weight from [;[ in the union H’, and (2) any balanced cut automatically has at least A
total weight from H.

1. For a mincut 0S* in G with volg(S*) < [0aS*|/¢ = /¢, the cut crosses

w(955") < vol - (S*) < O(1) - volg(S*) /A < O(1/¢)

edges in H, for a total cost of at most O(1/¢) - O(ed)) < e.
2. For a balanced cut 09, it satisfies [0gS| > ¢ - volg(S) > a, so it crosses

w(055) > O(1) - vol5(S) > O(1) - volg(S) /A > O(a/9)

many edges in H, for a total cost of at least ©(cr/¢) - O(eA). Setting a := O(1), the
cost becomes at least A.
Therefore, in the weighted graph H’, the mincut has weight at most (1 + O(e))\, and any
cut has weight at least (1 —e)\. We can reset € to be a constant factor smaller so that the
factor (1 + O(e€)) becomes (1 + €).
To finish the proof of Theorem [6.4.1} it remains to extract an unwelghted graph H and
a weight W from the weighted graph H’. Since W = @(ﬂ) =0(==* ¢’\) and W = O(ep)),

g g alogn logn
we can make W an integer multiple of IV, so that each edge in H " is an integer multiple of
W. We can therefore set W := W and define the unweighted graph H so that #py(u,v) =

wH/(u,v)//W for all u,v € V.

6.5 General Case

This section is dedicated to proving Theorem [6.3.4. For simplicity, we instead prove the
following restricted version first, which has the additional assumption that the maximum
edge weight in G is bounded. At the end of this section, we show why this assumption can
be removed to obtain the full Theorem [6.3.4

Theorem 6.5.1: Sparsifier with maximum weight assumption

There exists a function f(n) < 200esn)¥(oglogm)®® guch that the following holds.
Let G be a graph with mincut A\ and maximum edge weight at most ¢'\/f(n).
For any 0 < € < 1, we can compute, in deterministic 90(logn)*/%(loglog ) *™) 1y time, an
unweighted graph H and some weight W > €*\/f(n) such that the two properties of
Theorem hold, i.e.,

1. For any mincut S* of G, we have W - |0yS*| < (1 + ¢€)A, and

2. For any cut ) € S CV of G, we have W - |0y S| > (1 — €)\.
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6.5.1 Expander Decomposition Preliminaries

Our main tool in generalizing the expander case is expander decompositions, which was
popularized by Spielman and Teng [I00] and is quickly gaining traction in the area of fast
graph algorithms. The general approach to utilizing expander decompositions is as follows.
First, solve the case when the input graph is an expander, which we have done in Section
for the problem described in Theorem Then, for a general graph, decompose it into
a collection of expanders with few edges between the expanders, solve the problem each
expander separately, and combine the solutions together, which often involves a recursive
call on a graph that is a constant-factor smaller. For our purposes, we use a slightly stronger
variant than the usual expander decomposition that ensures boundary-linkedness, which will
be important in our analysis. The following definition is inspired by [45]; note that our
variant is weaker than the one in Definition 4.2 of [45] in that we only guarantee their
property (2). For completeness, we include a full proof in Section that is similar to the
one in [45].

Theorem 6.5.2: Boundary-linked expander decomposition, restated

Let G = (V, E) be a graph and let » > 1 be a parameter. There is a deterministic
algorithm in m!+°(/7) 4 O(m/¢?) time that, for any parameters 8 < (logn) 20" and
¢ < B, partitions V = V) W - - - WV such that

1. Each vertex set V; satisfies

@CSC% mln{VOIG[V]( )+ ’UJ(EG(S V\V)),VOIG[V](V\S)-FE?,U(EG V\S V\V)

Informally, we call the graph G[V;] together with its boundary edges Eq(V;, V\V;)
a [-boundary-linked ¢-expander.In particular, for any S satisfying

volgv; (S) + éw(EG(& VAV;)) < volg, (Vi \ S) + éw<EG(Vi \ S, V\ V),

¢ ¢
we simultaneously obtain
UJ(aG[V.]S) w(@G[V.]S) w(ag[v.]S)
— > and . > ¢ — : > 8.
volonn(3) = ¢ ™ Tu@as v\ = ¢ T wEBes, VA 2

The right-most inequality is where the name “boundary-linked” comes from.

2. The total weight of “inter-cluster” edges, w(dV; U --- U dV), is at most
(log n)° ") pvol (V).

Note that for our applications, it’s important that the boundary-linked parameter [ is
much larger than ¢. This is because in our recursive algorithm, the approximation factor
will blow up by roughly 1/ per recursion level, while the instance size shrinks by roughly
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0.

In order to capture recursion via expander decompositions, we now define a boundary-
linked expander decomposition sequence {G'} on the graph G in a similar way to [45]. Com-
pute a boundary-linked expander decomposition for g and ¢ < 8 to be determined later,
contract each expanderﬂ and recursively decompose the contracted graph until the graph
consists of a single vertex. Let G° = G be the original graph and G',G?,...,G* be the
recursive contracted graphs. Note that each graph G* has minimum degree at least ), since
any degree cut in any G induces a cut in the original graph G. Each time we contract, we
will keep edge identities for the edges that survive, so that E(G°) 2 E(G') 2 --- D E(G*F).
Let U* be the vertices of G*.

For the rest of Section , fix an expander decomposition sequence {G'} of G. For any
subset ) € S C V, we now define an decomposition sequence of S as follows. Let S = S,
and for each i > 0, construct S**! as a subset of the vertices of G**!, as follows. Take the
expander decomposition of G?, which partitions the vertices U of G* into, say, U?, ..., U,@Z
Each of the U ; gets contracted to a single vertex u; in G*. For each U }, we have a choice
whether to add u; to S* or not. This completes the construction of S°. Define the “difference”
Di = U;\ S"if u; € 5%, and D} = U; N S* otherwise. The sets S, U7, and D} define the
decomposition sequence of S.

We now prove some key properties of the boundary-linked expander decomposition se-
quence in the context of graph cuts, which we will use later on. First, regardless of the
choice whether to add each u; to S?, we have the following lemma relating the sets Dj- to
the original set S.

Lemma 6.5.3

For any decomposition sequence {S*} of S,

L
05 €| J | 9:Dj.

1=0j€[k;]

Proof. Observe that

(06:S") A0 S € | 06Dl (6.5)
]

jE€lks

In particular,
8@-5" - 8@i+15i+1 U U 8G1D;

J€lk]

1Since we are working with weighted multigraphs, we do not collapse parallel edges obtained from con-
traction into single edges.
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Iterating this over all 7,

L
05 €| J | 9a:Dj.

1=0 je[k;]

We now define a specific decomposition sequence of S, by setting up the rule whether or
not to include each u; in S*. For each U;, if

volgiy(S'NU;) + gw(Egi(SiﬂU;, U\U;)) = volgi (U;\ S) +§w(EGi(U;\Si, U\u)),

then add u; to S%; otherwise, do not add w; to S*. This ensures that
j j

volgis (U \ D3) + “w(Eg: (U} \ D}, U\ UY)) > volgiuy (D3) + “w(Eg: (D5, U\ U})).

¢ oo
(6.6)
Since G*'[U JZ] is a S-boundary-linked ¢-expander, by our construction, we have, for all i, j,

WewDy) (67)
VOlGi [UJZ] (Dj)

and

w(0gi [U}]D;‘)
w(Be (D, 0\ 07)) = (6:8)

For this specific construction of {S'}, called the canonical decomposition sequence of S,
we have the following lemma, which complements Lemma [6.5.3

Lemma 6.5.4

Let {S%} be any decomposition sequence of S satisfying for all 4, j. Then,

L
> w(0a D)) < B7Pw(06S).

=0 je[ki]

Proof. By ,

w(Eq: (D5, U\ U;)) < w(aGi[U;f]D;')‘

1
B
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The edges of Oz [U@]Dé are inside 0 S* and are disjoint over distinct j, so in total,
i 1 i 1 i
j€lki] J€lki]
From (/6.5]), we also obtain

8@“5”1 - 6(;¢Si U U angl

-
Jj€lki]
Therefore,
w(ﬁgi+152+1) < w(agz‘5ﬂ> +w U 8G1D1 < (1 + E) . w(agiSZ).
JE[Ki]

[terating this over all ¢ € [L], we obtain

w(9:S') < (1 + %) - w(06S).
Thus
L L 1 L 1 )
> weD; ZB w(dg:SH) <Y 5 ( —> w(06S) = B~ w(96S).
i=0 je[k] i=0 i=0

6.5.2 Unbalanced Case

In this section, we generalize the notion of unbalanced from Section to the general case,
and then prove a (1 + €)-approximate sparsifier of the unbalanced cuts.

Fix an expander decomposition sequence {G'} of G for the Section m For a given set
P C SV, let {S'} be the canonical decomposition sequence of S, and define D’ as before,
so that they satisfy and for all 7,7. We generalize our definition of unbalanced

from the expander case as follows, for some 7 = n°M) to be specified later.

Definition 6.5.5

The set S C V is T-unbalanced if for each level ¢, 3, | volgi (D) < 7A/é. A cut OS
is 7-unbalanced if the set S is 7-unbalanced.

Note that if G is originally an expander, then in the first expander decomposition of the
sequence, we can declare the entire graph as a single expander; in this case, the expander de-
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composition sequence stops immediately, and the definition of 7-unbalanced becomes equiv-

alent to that from the expander case. We now claim that for an appropriate value of 7, any
mincut is 7-unbalanced.

For 7 > =) any mincut 9S* of G is T-unbalanced.

Proof. Consider the canonical decomposition sequence of S, and define D; as usual. For
each level i and index j € [k;],

volg: (D)) = volgiis (Dj) + w(Ee:(Dj, U\ Uj))

-1 i i 77i i
1
¢ w(0: D).
Summing over all j € [k;] and applying Lemma [6.5.4]
) 1 _ Lem[5E54 ] A
S volgi (D) < 37 Swl@eDl) = - 3 wloeD)) ST S 5 OWu(0psT) < 7
o — ¢ o _ ¢ ¢
JElki] J€Elki JE[ki]
so S* is T-unbalanced. O

Let us now introduce some notation exclusive to this section. For each vertex v € U?, let
v C V be its “pullback” on the original set V', defined as all vertices in V' that get contracted
into v in graph G* in the expander sequence. For each set Dz let D’ C V be the pullback

of D?, defined as D;. = Upe pi U We can then write

Sy Y e

7] ’UEDZ

where the £ sign depends on whether D = U\ S* or D} = U N S*. Then,

w(06S) = 1§Lalsg = » | +1%- Lalpy = YooY #1lLels (6.9)

’.7 k l ivjﬂkﬂl ’UIGD;,’UED{C

For an 7-unbalanced set S, there are at most ((L + 1)7/¢)? nonzero terms in the

summation .

Proof. Each vertex v € Dj- has degree at least \ in G, since it induces a cut (specifically, its
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pullback ¥ C V') in the original graph G. Therefore,

TA/ > ) vola(Di) > Y |Di|-

JE[ki] j€[ki)

so there are at most 7/¢ many choices for j and u € D} given a level 7. There are at most
L + 1 many choices for i, giving at most (L + 1)7/¢ many combinations of i, j, u. The same
holds for combinations of k, [, v, hence the claim. O

The main goal of this section is to prove the following lemma.

Lemma 6.5.8

Cep
7In(Lm)?

compute, in deterministic O(L?m) timeEI an unweighted graph H such that for all
levels i, k and vertices u € U',v € U* satisfying degq:(u) < 7A/¢ and degqu(v) <

A/,

There exists a constant C' > 0 such that given any weight W < we can

|12 Laly — W - 12 Lylg| < €. (6.10)

®outside of computing the boundary-linked expander decomposition sequence

Before we prove Lemma [6.5.8, we show that it implies a sparsifier of 7-unbalanced cuts,
which is the lemma we will eventually use to prove Theorem [6.5.1

Lemma 6.5.9

Cep
7In(Lm)’

compute, in deterministic O(L2m) time, an unweighted graph H such that for each

There exists a constant C' > 0 such that given any weight W < we can

T-unbalanced cut S,

Proof. Let C > 0 be the same constant as the one in Lemma [6.5.8. Applying to Oy S
as well, we have

w(0eS) =W -w@uS) =Y >  £(1ELgly— W - 11Lyly),

L3kl ue D} weDF

so that
w(0eS) =W -w(@u8)| <> Y |1ELgly =W - 15 Lyl

3kl ue Dl weDF

By Claim [6.5.7] there are at most ((L + 1)7/¢)? nonzero terms in the summation above. In
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order to apply Lemma to each such term, we need to show that degq:(u) < 7A/¢ and
deger(v) < T7A/¢p. Since S is an T-unbalanced cut, we have

deggi(u) < volg:(D}) < Z volg:i (D)) < TA/¢,
J€lki]

and similarly for degg(v). Therefore, by Lemma[6.5.8]

|w0(06:S) — W - w(0sS)| < (W) Lo\,

as desired. ]

The rest of Section is dedicated to proving Lemma

Expand out Lg = ) .5 Le, where L, is the Laplacian of the graph consisting of the
single edge e of the same weight, so that 12 L 15 € {—w(e),w(e)} if exactly one endpoint of
e is in u and exactly one endpoint of e is in 7, and H%Le 1y = 0 otherwise. Let Ey5 4 denote
the edges e € F with 1L, 15 = w(e), and Fz5 _ denote those with 1L, 1; = —w(e).

Random Sampling Procedure Consider the Benzcur-Karger random sampling proce-
dure, which we will de-randomize in this section. Let H be a subgraph of G with each
edge e € E sampled independently with probability w(e)/W, which is at most 1 by the
assumption of Theorem Intuitively, the parameter W > A/ f(n) is selected so that
with probability close to 1, holds over all i, k, u, v.

We now introduce our concentration bounds for the random sampling procedure, namely
the classical multiplicative Chernoff bound. We state a form that includes bounds on the
moment-generating function E[e'¥] obtained in the standard proof.

Lemma 6.5.10: Multiplicative Chernoff bound

Let Xi,..., Xn be independent random variables that take values in [0, 1], and let
X =" X;and p=E[X] = ¥, pi. Fix a parameter §, and define
u l 1
Then, we have the following upper and lower tail bounds:
Pr[X > (14 0)u] < e "OHIRE[eHX] < =04/3, (6.12)
Pr[X < (1 — 8)p] < ' -OE[etX] < ¢ 04/3, (6.13)

We now describe our de-randomization by pessimistic estimators. Let F' C E be the set
of edges for which a value X, € {0,1} has already been set, so that F is initially (). For
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each i, k, vertices u € U',v € U*, and sign o € {+, —} such that E;5, # 0, we first define
a “local” pessimistic estimator ®z7.(+), which is a function on the set of pairs (e, X.) over
all ¢ € F. The algorithm computes a 3-approximation A € [X,3A] to the mincut with the
O(m)-time (2 + €)-approximation algorithm of Matula [80], and sets

w(Eﬂgo) GX
o = T d Oupo = 0~ 6.14
’Uy W arn »Uy Gw(Eﬂ7§’o> ( )
Following (6.11]), we define
u ! 1
tizo =In(1+0ups)  and  f;,=1In 1— 0350/ (6.15)

and following the middle expressions (the moment-generating functions) in (6.12)) and (6.13]),
we define

Punol{(e, X0) s e € F}) = e thoalttounamne T eliuee ] Efetne)

eEEg,g,oﬂF eeEﬂ’g’o\F
! l l
+ etﬁj,o(l_éij,o)l"ﬁ,i,o H e_tij,oX€ H E[e_tgije] .
GEEH,§70 NF €€Eﬁ’§’O\F

Observe that if we are setting the value of X,/ for a new edge €’ € Egz5,\ F, then by linearity
of expectation, there is an assignment X, € {0, 1} for which @5 .(-) does not decrease:

Pugo({(e, Xe) e € FYU (e, X)) < Pumo({(e, Xe) s € € F}).

Since the X, terms are independent, we have that for any t € R and E' C F,

E [etZeeEl XE} — H ]E[etxe].

ecE’!

By the independence above and the second inequalities in (6.12)) and (6.13), the initial “local”
pessimistic estimator @5 ,(() satisfies

5uu) e (_(EA/(ﬁw(EM,O))V w(Eazo) /W.>

(I)HEO S 2 -
w.0(0) eXp( 3 3

=2exp | — 2%
— P T80 (B )

We would like the above expression to be less than 1. To upper bound w(Ey3,), note first
that every edge e € Fy5,. must, under the contraction from G all the way to G*, map to an
edge incident to u in G, which gives w(Ey3.) < deggi(u). Moreover, since degg:(u) < T7A/¢

100



by assumption, we have
w(Eppo) < deggi(u) < 7A/¢ (6.16)

so that

N2 €2 B EPA
Porell) = 2o <_W> <20 (~imaran ) =200 (~imeaw)

Assume that

EGA
W <
~ 1087 In (16(L + 1)?m)’

which satisfies the bounds in Lemma [6.5.8], so that

EOA 1
(e <2 — < .
wve(0) < eXp( 1087W) = 8L+ 1)2m

Our actual, “global” pessimistic estimator ®(-) is simply the sum of the “local” pessimistic
estimators:

d({(e,X.):e€ F}) = Z Duso({(e, X.) : e € F}).

weUtwelk,
OE{—‘,—,—}

The initial pessimistic estimator ®(()) satisfies

1 ClmE5.12 1 1
o)) = D 50(0) < — < AL+ 1D)Pm— =,
@) ; woll) = ; 8(L+1)>m  — (L+1)m 8(L+1)2m 2
uEUi:v:EUk, ueUi:v%Uk,
oe{+,—} oe{+,—}

Again, if we are setting the value of Xy for a new edge f € E \ F, then by linearity of
expectation, there is an assignment Xy € {0, 1} for which ®(-) does not decrease:

O({(e,X.) e € FYU(f, X)) < ®({(e, X.) : e € F}).

Therefore, if we always select such an assignment X., then once we have iterated over all
e € E/, we have

d({(e; Xe)re€ B}) <0(0) <5 <L (6.18)
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This means that for each i, k,u € U*,v € U, and sign o € {+, -},

bazo({(e; Xe) : e € E})
— ¢tz (IH0umo)tmm,o H etimoXe 4 otnpo(l=dmmo)imm,o H e tamoXe < 1.

e€EFg 50 e€lby 5.0

In particular, each of the two terms is at most 1. Recalling from definition (6.14) that
,uﬂ,@,o = w(Eﬂj’o)/W and 55,@70 = €A/(6/LU(EE’§70)), we have

w(Eﬂﬁo> GX
X, < (14 0upo)ituno = o,
> < (L+ damo)pag, + &7
EeEiygyc
and )
w(Eﬂﬁo) €A
X, > (1= 6gp0)flmpe = wo) _ A
Z - ( 'y )ILL s Uy W 6W
EEEE,E,O
Therefore,
Lot —WtlLgtel < Y ulBen) - W- Y X< DA Doy
u u H — Oe{+ _} 'Yy e€E77 -~ 6 6 3 < s

fulfilling (6.10)).

It remains to consider the running time. We first bound the number of 7, k, u, v such that
either Fyp 4 # 0 or Eyp_ # 0; the others are irrelevant since 12 Lgly = 121 ily = 0.

For each pair of vertices z,y, there are at most (L + 1) many selections of 4, k and
u € U',v € U* such that x € w and y € T.

Proof. For each level 4, there is exactly one vertex v € U’ with 2 € @, and for each level
k, there is exactly one vertex v € U* with y € v. This makes (L + 1)? many choices of i, k
total, and unique choices for u, v given ¢, k. O

For each edge e € F, there are at most 4(L+1)? many selections of i, k and u € U%,v €
Uk such that e - Eﬁj’_i_ U Eﬂ’57_.

Proof. 1t e € Ey5 .4+ U Eq5 —, then exactly one endpoint of e is in % and exactly one endpoint
of e is in v. There are four possibilities as to which endpoint is in w and which is in v, and

for each, Claim [6.5.11| gives at most (L + 1)? choices. O
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There are at most 4(L + 1)?>m many choices of 4, k, u, v such that either Fy5 . # 0 or

Fus #0.

Proof. For each such choice, charge it to an arbitrary edge (z,y) € Fyz4 U Eg@ Each

edge is charged at most 4(L + 1)? times by Claim [6.5.12] giving at most 4(L + 1)%*m total
charges. O

By Claim [6.5.12] each new edge e € E'\ F is in at most 4(L + 1)® many sets Eyz,, and
therefore affects at most 4(L + 1)? many terms ®5.({(e, X.) : € € F'}). The algorithm only
needs to re-evaluate these terms with the new variable X, set to 0 and with it set to 1, and
take the one with the smaller new ®(-). This takes O(L?) arithmetic operations.

How long do the arithmetic operations take? We compute each exponential in ®(-)
with clogn bits of precision after the decimal point for some constant ¢ > 0, which takes
polylog(n) time. Each one introduces an additive error of 1/n°, and there are poly(n)
exponential computations overall, for a total of 1/n¢- poly(n) < 1/2 error for a large enough
¢ > 0. Factoring in this error, the inequality instead becomes

S({(e.X.) e € EY) <®) + <411,

[\
[N}
[\

so the rest of the bounds still hold.
This concludes the proof of Lemma [6.5.8|

6.5.3 Balanced Case

Similar to the expander case, we treat balanced cuts by “overlaying” a “lossy”,
n°M_approximate sparsifier of G' top of the graph H obtained from Lemma . In the
expander case, this sparsifier was just another expander, but for general graphs, we need
to do more work. At a high level, we compute an expander decomposition sequence, and
on each level, we replace each of the expanders with a fixed expander (like in the expander
case).

Theorem 6.5.14: Lossy cut sparsifier

Let G be an weighted multigraph with mincut A whose edges have weight at most O()).
For any parameters A € [\, 3)] and A > 2008m” e can compute, in deterministic
90(logn)*/®(loglogm)®™Myy 4 O (Am) time, an unwelghted multigraph H such that W - H
is a y-approximate cut sparsifier of G, where v < 90(logn)*/%(loglog )™ 514 W = X/ A.
(The graph H does not need to be a subgraph of G.) Moreover, the algorithm does
not need to know the mincut value .

103



6.5.4 Combining Them Together

We now combine the unbalanced and balanced cases to prove Theorem Our high-level
procedure is similar to the one from the expander case. For the 7-unbalanced cuts, we use
Lemma [6.5.9] For the balanced cuts, we show that their size must be much larger than A, so
that even on a y-approximate weighted sparsifier guaranteed by Theorem[6.5.14] their weight
is still much larger than A\. We then “overlay” the y-approximate weighted sparsifier with a
“light” enough weight onto the sparsifier of 7-unbalanced cuts. The weight is light enough
to barely affect the mincuts, but still large enough to force any balanced cut to increase by
at least X\ in weight.

If a cut S is balanced, then w(9gS) > BT\

Proof. Consider the level i for which }_ ., , volgi(D}) > 7A/¢. For cach j € [k;], we have

volg:(D}) = volgi (D)) + w(Eg: (D}, U\ U})) < ; (O D) + w(Ee: (Dy, U\ U)
< glb ( (3Gi[U;i]D§) +w(Ei(D}, U\ UJZ))>
~ (00D},
so summing over all j € [k;],
Z %w(aGiD;i) > Y volgi(Di) > %

J€ k]
By Lemma [6.5.4] it follows that

(aGS > BO L) Z az Dz > BO(L

JElki]
O

We now set some of our parameters; see Figure [6.1] for a complete table of the parameters
in our proof. For r := (logn)%, let B := (logn)=°"") and ¢ := (logn)™"", so that by
Theorem the total weight of inter-cluster edges, and therefore the total weight of
the next graph in the expander decomposition sequence, shrinks by factor (log n)o(’A)d) =
(log n)_Q(’j). Since edge weights are assumed to be polynomially bounded, this shrinking
can only happen O(*%%") times, so L < O(*%5%).

Let X € [\, 3\ be a 3-approximation to the mincut, computable in O(m) time [80],
Let € := ( )2e for parameter 7 that we set later, and let H be the sparsifier of 7-

2
(L+1)7
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Par.| Value
Mincut of G

3-approximation of A

Given as input

(log )/

(logn) 90" from Theorem |6.5.2|
)

o

(logn)~
O(lorgsn)
20(logn)? % (loglog n) °™ from Theorem |6.5.14|
20(10gn)*® from Theorem |6.5.14|

B=¢L~2 /e for large enough constant ¢ > 0
1( ¢ )26
(L+1)T
Ce' oA
L7 In(Lm)

N>R e | >

(@)}
~

min{ , A} where C' > 0 is the constant from Lemma|6.5.9

S| =)

€ .

2y

D>

Figure 6.1: The parameters in the proof of Theorem [6.5.1]

unbalanced cuts from Lemma [6.5.9) [6.5.9] for this value of ¢ (instead of €) and the following value

of W < 06( ‘z”\) (taking the place of W):

T d Ceed A e\ A
e 3rin(Lm)” A i L2 In(Lm) | A [

Let H be the unweighted graph from Theorem |6.5.14] applied to X and A, so that A JA- Hisa
. Define I := £ - %, and let H' be

the “union” of the graph H weighted by W and the graph H weighted by w. More formally,
consider a weighted graph H' where each edge (u, v) is weighted by W-wz(u, v)+W-wgz(u,v).

~-approximate cut sparsifier for ~ := 20(osn)*/®(loglogn)

For an 7-unbalanced cut 05, the addition of the graph H weighted by W increases its

Weighl by
w H w H Yyw G w G 5

so that
‘w(&GS) . (W w(058) + W - w(8g5)> ] < [w(06S) — W - w(@58)| + W - w(055")
EX €

= ? + 2?1)(8@5)

< ew(0aS).
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In particular, any 7-unbalanced cut satisfies
(1= A< W - w(058) + W - w(055) < (1+€)A. (6.19)

Next, we show that all balanced cuts have weight at least A in the graph H weighted
by W. This is where we finally set 7 := 87°t~2/e for large enough constant ¢ > 0. For a
balanced cut S,

ClmB515 ¢

. A 1
W - w(07S) = % <Zw(ag5)) > % (;w(agso > " BTN > A,

Moreover, by Claim for this value of 7 > =9 the mincut 0S* is T-unbalanced, and

therefore has weight at least (1 — €)X\ in H' by (6.19)).
Therefore, H' preserves the mincut up to factor € and has mincut at least (1 —€)\. It

remains to make all edge weights the same on this sparsifier. Since W = % . % and the

only requirement for A from Theorem [6.5.14 is that /\Ag 20(1°g")5/6, we can increase or
decrease A by a constant factor until either W /W or W /W is an integer. Then, we can let
W = min{W, W} and define the unweighted graph H so that #g(u,v) = wgy/(u,v)/W for

all u,v € V. Therefore, our final weight W is
—~ —~ engSX N e
W =min{W, W} =min Q| —+—7-—1|,—<, — -
min{W, W} mln{ <T3L2 ln(Lm)) TAT 2y }
so we can set f(n) := 200osn)?*(loglogm)®Y " 4¢ Jegired.
Finally, we bound the running time. The expander decomposition sequence (Theo-

O(l))\’
rem [6.5.2) takes time m!t1/") 4 O(m/¢?), the unbalanced case (Theorem [6.5.2) takes
time O(L?m), and the balanced case takes time 200°8 n)?/8(loglogm)®™ - Altogether, the total
js 20(logn)*/(loglog ")O(l)m, which concludes the proof of Theorem .

L] >t

> 64270(10g n)5/6(log log n))

6.5.5 Removing the Maximum Weight Assumption

Let f(n) = 200cgn)*®oglogm)®™ ho the function from Theorem [6.5.1] In this section, we
show how to use Theorem [6.5.1] which assumes that the maximum edge weight in G is at
most e*\/f(n), to prove Theorem which makes no assumption on edge weights.

First, we show that we can assume without loss of generality that the maximum edge
weight in G is at most 3A. To see why, the algorithm can first compute a 3-approximation
X € [\, 3)] to the mincut with the O(m)-time (2-+¢)-approximation algorithm of Matula [0,
and for each edge in G with weight more than X, reduce its weight to X. Let the resulting
graph be G. We now claim the following:
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Suppose an unwelghted graph H and some weight W satisfy the two properties of
Theorem 4 for G. Then, they also satisfy the two properties of Theorem 4 for
G.

Proof. The only cuts that change value between G and G are those with an edge of weight
more than X, which means their value must be greater than A> A In particular, since G
and G have the same mincuts and the same mincut values, both properties of Theorem
also hold when the input graph is G. O

For the rest of the proof, we work with G instead of G. Define W := €4)\/(3f(n)), which
satisfies W < €')/f(n). For each edge e in G, split it into [w(e)/W] parallel edges of weight
at most W each whose sum of weights equals w(e); let the resulting graph be G. Apply
Theorem on G, which returns an unweighted graph H and weight W > e*\/ f(n) such
that the two properties of Theorem “ 4| hold for G. Clearly, the cuts are the same in G
and G: we have w(055) = w(0sS) for all S C V. Therefore, the two properties also hold
for @, as desired.

We now bound the size of G and the running time. Since w(e) < A, we have [w(e)/W] <
[3f(n)/€'], so each edge splits into at most O(f(n)/e*) edges and the total number of
edges is m < O(f(n)/€*) - m. Therefore, Theorem [6.5.1] takes time 20(cs™)*/*(loglogn)®® 7
¢—420(logn)*/%(log log ")O(l)m, concluding the proof of Theorem M

6.6 Conclusion

In this chapter, we presented a deterministic, almost-linear time algorithm for global mincut.
One immediate open question is whether the running time can be improved to m polylog(n)
to match the randomized complexity up to polylog(n) factors. This direction has two signifi-
cant obstacles, however. The first, which is discussed in Section [8.9} is that the deterministic
expander decomposition algorithm already takes m!'*t°(®) time, and improving even that to
mpolylog(n) would require significantly new ideas. Moreover, even if that were accom-
plished, the nature of our boundary-linked expander decomposition hierarchy would still in-
cur an additional 20(Vlesnloglogn) factor, so the overall running time is still m20(vicgnloglogn)
Bypassing the expander decomposition hierarchy would itself require novel ideas that may
see applications to other graph cut problems.
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Chapter 7

Parallel Shortest Path

In this chapter, we discuss our preconditioning-based approach to computing approximate
shortest paths in parallel. Our main result is a parallel algorithm to compute (1 + €)-
approximate single-source shortest paths in m polylog(n) work and polylog(n) time, based
on the work of [6§].

We approach this problem from a continuous perspective by studying the closely re-
lated minimum transshipment problem, which we view as a continuous relaxation of the
single-source shortest paths problem. To solve minimum transshipment, we combine precon-
ditioning with iterative methods that minimize continuous functions in a small number of
parallel rounds. Notably, our approach deviates from the previous hopset-based shortest path
algorithms which come close to, but do not quite attain, the targeted m polylog(n) work and
polylog(n) time. In other words, this chapter serves as evidence that preconditioning-based
methods, when combined with the inherent parallelism of iterative methods, is a promising
research direction in parallel graph algorithms.

Our algorithm for (1 4+ e€)-approximate minimum transshipment follows the
preconditioning-based framework of Sherman [97]. In the context of graph distance algo-
rithms, the well-conditioned graphs are precisely the low-diameter graphs, and Sherman’s
key insight is that there is a simple transshipment algorithm on such graphs. To generalize
the algorithm to all instances, we compute low-diameter decompositions—the distance-based
equivalent of expander decompositions—at varying diameter scales. To control the errors
over all scales, we actually compute the low-diameter decompositions on an embedding of the
graph into high-dimensional Euclidean space. This part of our algorithm improves upon Sher-
man’s original transshipment algorithm and is required to achieve the desired m polylog(n)
work and polylog(n) time. Finally, as Sherman’s algorithm only works for the sequential
setting, we face new challenges in developing a fully parallel algorithm. We end up adopting
a recursive framework, reducing the minimum transshipment problem to sufficiently smaller
instances of itself.
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7.1 Background

The single-source shortest path problem is one of the most fundamental combinatorial op-
timization problems, and is also among the most notorious in parallel computation models.
While the sequential model has simple near-linear time algorithm dating back to Dijkstra,
much remains unknown for even the PRAM model despite decades of extensive research.

One of the most well-known settings studied so far in the PRAM model is the case of
(1 + €)-approximate single-source shortest paths in undirected graphs. Early work on this
problem produced algorithms in sublinear time [61, [62], until the breakthrough result of
Cohen [28], who presented an algorithm in O(m'*%) work (for any constant ¢y > 0) and
polylog(n) time through the use of hopsets: additional edges added to the graph so that
short paths in the graph span few edges. Since then, it was a long-standing open problem
whether Cohen’s algorithm could be improved to run in m polylog(n) work while keeping
the time polylog(n).

Recently, this question was partially answered by Abboud, Bodwin and Pettie [I], surpris-
ingly in the negative: they showed that there exist families of graphs for which any hopsets
on these graphs must have size Q(m!*<), thereby lower bounding the work by Q(m!T<) for
any purely hopset-based algorithm like Cohen’s. While their lower bound does not rule out
other approaches to this problem, no other directions of attack have come close to matching
Cohen’s method of hopsets before the results of this chapter and the concurrent work of
Andoni, Stein, and Zhong [9] on the same problem.

7.1.1 Our Contributions

In this chapter, we tackle this problem from a new perspective: continuous optimization,
especially the methods pioneered by Sherman [96] for the max-flow problem. By reducing
to studying the closely-related and more continuous minimum transshipment problem, we
provide the first (1 + €)-approximate SSSP algorithm for weighted, undirected graphs in
m polylog(n) work and polylog(n) time in the PRAM model, bypassing the hopset lower
bound and resolving the aforementioned open problem. This serves as evidence that con-
tinuous optimization, with its rich theory in graph algorithm and inherent parallelism, is a
promising research direction in parallel graph algorithms and can bypass known barriers to
other common approaches.

Theorem 7.1.1: Parallel SSSP

There exists a parallel algorithm that, given an undirected graph with nonnega-
tive weights, computes a (1 + €)-approximate single-source shortest path tree in
mpolylog(n) e~? work and polylog(n) e~2 time in the PRAM model.

Our SSSP algorithm is recursive, cycling through three problems in a round-robin fash-
ion: SSSP, transshipment, and the problem of computing an /;-embedding of a graph with
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polylog(n) distortion in O(logn) dimensions. That is, each problem calls the next problem
on the cyclic list possibly many times, and possibly on a smaller graph instance. Hence, we
obtain parallel algorithms with similar running times for the other two problems as well.

Theorem 7.1.2: Parallel transshipment

There exists a parallel algorithm that, given an undirected graph with nonnegative
weights and polynomial aspect ratio, computes a (1 + €)-approximation to minimum
transshipment in m polylog(n) €2 work and polylog(n) €2 time in the PRAM model.

Theorem 7.1.3: Parallel /;-embedding

There exists a parallel algorithm that, given an undirected graph with nonnegative
weights and polynomial aspect ratio, computes an ¢;-embedding with polylog(n) dis-
tortion in O(log n) dimensions in m polylog(n) work and polylog(n) time in the PRAM
model.

Theorem also establishes the first m polylog(n) time sequential algorithm for (1+¢)-
approximate transshipment, improving upon the m!*°-time algorithm of Sherman [97]. For
readers primarily interested in the sequential setting, we further optimize our parameters to
the following. Note that the best algorithm for the closely-related max-flow problem [90)]
requires O(mlog* n) time in comparison. Our algorithm is also technically considerably
simpler than the max-flow algorithm, and may serve as a gentler introduction to readers
new to continuous optimization methods in graph algorithms.

Theorem 7.1.4: Sequential transshipment

There is an algorithm that, given an undirected graph with nonnegative weights and
polynomial aspect ratio, computes a (1+ €)-approximation to minimum transshipment
in time O((mlog' n + nlog' n) e2 (loglogn)°W).

7.1.2 Owur Techniques

We follow Sherman’s preconditioning-based approach for transshipment [97|. Sherman’s
framework reduces the problem of approximate transshipment to that of ¢1-oblivious routing
in a matrix-theoretic sense. More precisely, the task is to compute a sparse matrix R such
that for any transshipment demand vector b, the value ||Rb||,; approximates up to polylog-
arithmic factors the minimum transshipment cost with demands b. Note that if the graph
has aspect ratio A, then setting R as the square identity matrix approximates the minimum
transshipment cost up to factor 2A. (To see this, first consider the case when b = 1, — 1,
and then decompose a general demand into such vectors according to the minimum trans-
shipment flow.) In other words, for average-case instances with low aspect ratio, computing
a good matrix R is trivial. To handle general graphs, we first compute an ¢;-embedding
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and essentially phrase the question purely from a geometric point of view, which was also
Sherman’s approach [97]. We then decompose the embedded vertices into grid cells, which
can be viewed as a graph distance analogue of expander decomposition. Our key new in-
sight is to randomly shift the grid when building the grid cells, a technique borrowed from
low-dimensional computational geometry [49]. The ¢;-oblivious routing algorithm is mostly
self-contained and has no relation to the parallel sections of the chapter. We therefore iso-
late it in its own section, Section [7.4], for the convenience of readers primarily interested in
transshipment in the sequential setting.

Our recursive algorithm is inspired by a similar recursive algorithm by Peng [90] for
max-flow. It is instructive to compare our result to that of Peng [90], the first O(m)
timeﬂ algorithm for (1 — e)-approximate max—ﬂowE] Peng [90] uses an oblivious routing
scheme for max-flow that achieves polylog(n)-approximation, but requires polylog(n) calls
to (1 — €)-max-flow [91]. This oblivious routing scheme produced a chicken-and-egg situa-
tion for max-flow and oblivious routing, since each one required calls to the other. Peng’s
main contribution is breaking this cycle, by allowing the oblivious routing to call max-flow
on sufficiently smaller-sized graphs to produce an efficient recursive algorithm. Here, we
adopt a similar recursive approach, cycling through the problems of shortest path, minimum
transshipment, oblivious routing, and ¢;-embedding.

Step 1: reduce to transshipment. The first step of the algorithm is to reduce the
approximate SSSP problem to the approximate minimum transshipment problem, which
was previously done in [14] for various other computational models. Making it work in the
PRAM model requires a little more care, and for completeness, we provide a self-contained
reduction in Sections [Z.8 and [7.9l

Note that if we were in the exact case, then the reduction would be immediate: there is
a straightforward reduction from exact SSSP to exact transshipment: set —(n — 1) demand
on the source vertex and +1 demand on the rest, and from the transshipment flow we can
recover the exact SSSP relatively easily. However, in the approximate case, an approximate
transshipment solution in the same reduction only satisfies distances on “average”. [14]
handles this issue through O(logn) calls to approximate transshipment with carefully and
adaptively constructed demands on each call; we use O(log n) calls instead with a more
sophisticated reduction.

Step 2: /;-oblivious routing. As mentioned before, Sherman’s framework reduces the
problem of approximate transshipment to that of ¢;-oblivious routing. We follow the same

!'Throughout this chapter, we use the standard 0() notation to hide polylogarithmic factors in the
running time.

2Note that max-flow and minimum transshipment are closely related: for graph incidence matrix A and
a diagonal matrix C' capturing the edge capacities/costs, and for a given demand vector b, the max-flow
problem is equivalent to min || f||, subject to Af = b, and the minimum transshipment problem is exactly
min 1 f subject to Af =b.
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approach in Section computing an ¢;-embedding matrix R given an initial /;-embedding
of the graph with polylog(n) distortion.

Step 3: /;-embedding and ultra-sparsification. Unfortunately, while ¢;-embeddings
are simple to compute sequentially, no work-efficient parallel algorithm is known. This is
because the popular algorithms that compute ¢;-embeddings sequentially all require distance
computations as subroutines, and no work-efficient parallel algorithm for SSSP is known. (If
one were known, then there would be no need for our result in the first place!)

Recall that we sought out to solve SSSP, and currently, our ¢;-embedding problem requires
an SSSP routine on its own. This is where Peng’s key insight comes to play: while recursing
naively on the same graph will not work (since it would loop endlessly), if we can recurse
on sufficiently smaller graphs, then the recursion analysis would produce an algorithm with
the desired running time. This is indeed Peng’s approach for max-flow: he makes one max-
flow instance call ¢ -oblivious routing, which in turn calls max-flow a number of times, but
ensures that the total size of the recursive calls is at most half the size of the original graph.
The recursion then works out to roughly T'(m) = 3. T(m;) + O(m) where 3. m; < m/2,
which solves to T'(m) = O(m).

How does Peng achieve the reduction in size? Instead of computing /..-oblivious routing
in the original graph G, he first (edge-)sparsifies G into a graph H on n vertices and (n—1)+
O(ﬁog(n)) edges by computing an ultra-sparsifier of the graph [63]. This is a graph that is
so sparse that it is almost “tree-like” (at least when m = O(n)). Of course, this alone might
not achieve the desired size reduction, for example if m ~ n. Therefore, he next vertex-
sparsifies H into a graph H' with O(ﬁog(n)) vertices and O(ﬁog(n)) edges using a j-tree
construction of Madry [77]. He now calls {,-oblivious routing on H’ (instead of &), which
again calls max-flow, but this time on graphs of small enough size (w.r.t. the original graph)
to make the recursion work out. Moreover, by the properties of the ultra-sparsifier and
the vertex-sparsifier, a polylog(n)-approximate ¢..-oblivious routing scheme for H’ is also
a polylog(n)-approximate /. .-oblivious routing scheme for G (that is, the approximation
suffers an extra polylog(n) factor). The specific polylog(n) factor does not matter at the
end, since in Sherman’s framework, any polylog(n) factor is sufficient to boost the error to
(1 + €) for max-flow at an additional additive cost of O(m).

Our approach is similar, but adapted from /., /max-flow to ¢;/transshipment. The /¢;-
analogy of an ultrasparsifier has been studied previously by Elkin and Neiman [34], who
coined the term wultra-sparse spanner; in this chapter, we will use ultra-spanner instead to
emphasize its connection to ultra-sparsifiers. Instead of running ¢;-embedding on G, we
compute an ultra-spanner H, and then vertex-sparsify it in the same manner as Peng; again,
the resulting graph H' has O(#Og(n)) vertices and edges. We then run ¢;-embedding on
H’, making calls to (approximate) SSSP on graphs of much smaller size. It turns out that
approximate SSSP works for the /;-embedding algorithm that we use, provided that the
distances satisfy a certain triangle inequality condition that our SSSP algorithm obtains for
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free.

7.1.3 Chapter Organization

In Section [7.3] we introduce the high-level components of our recursive parallel algorithm
(see Figure , leaving the details to later sections and the appendix.

Section[7.4)is focused exclusively on the sequential transshipment result (Theorem [7.1.4)).
The algorithm is almost completely self-contained, save for Sherman’s framework and an
initial /;-embedding step (which can be computed quickly sequentially [74]). It has nothing
deferred to the appendix in an attempt to make it a standalone section for readers primarily
interested in Theorem [7.1.4]

7.2 Additional Preliminaries

All graphs in this chapter are undirected and (positively) weighted, with the exception of
Section [7.8] where directed graphs and edges of zero weights are defined explicitly. For two
vertices u,v € V(G), we define dg(u,v) as the (weighted) distance between u and v in Gj if
the graph G is clear from context, we sometimes use d(u,v) instead.

7.2.1 PRAM Model

Our PRAM model is based off of the one in [37], also called the work-span model. An
algorithm in the PRAM model proceeds identically to a sequential algorithm except for the
addition of the parallel foreach loop. In a parallel foreach, each iteration of the loop must
run independently of the other tasks, and the parallel algorithm may execute all iterations in
parallel instead of sequentially. The work of a PRAM algorithm is the same as the sequential
running time if each parallel foreach was executed sequentially instead. To determine the time
of the algorithm, for every parallel foreach, we calculate the maximum sequential running
time over all iterations of the loop, and sum this quantity over all parallel foreach loops.
We then add onto the total the sequential running time outside the parallel foreach loops
to determine the total time. There are different variants of the PRAM model, such as
the binary-forking model and the unlimited forking model, that may introduce additional
overhead in foreach loops. However, these all differ by at most polylogarithmic factors in
their work and span, which we always hide behind O() notation, so we do not concern
ourselves with the specific model.

7.2.2 Transshipment Preliminaries

The definitions below are central for our sequential transshipment algorithm (Theorem [7.1.4]
Section and are also relevant for the parallel algorithms.
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Reduce SSSP to TS [14]

Algorithm [TS-to-SSSP| Section

O(logn) calls

Vertex reduction [77]

Sherman’s
Lemma [7.3.15
Section [7.5) framework [96]
Theorem [7.4.2]
call to SSSP(O(%-), O(%=), o) Section [.7]
og? n log n’/’ logn
Bourgain’s
embedding [74] O(log?n) calls ew (q-oblivious
Lemma [7.3.12 ) rOUtm% )
Section ’ maln contri utlon)
Section [7.4]
1 call

Ultra-sparsify
Lemma [7.3.14] Section

Figure 7.1: Our recursive approach, inspired by [90]’s for max-flow. SSSP(n,m, €) is the work
required to compute (1+¢)-approximate SSSP (on a graph with n vertices and m edges) that
satisfies a certain triangle inequality condition that we omit here. TS(n,m,€) is the work
required to compute (1 + €)-approximate transshipment. OR(n,m) is the work required
to compute a polylog(n)-approximate ¢;-oblivious routing (matrix), and LE(n,m) is the
work required to compute an ¢;-embedding in O(logn) dimensions with at most polylog(n)
distortion.
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Definition 7.2.1: Transshipment

The minimum transshipment problem inputs a (positively) weighted, undirected graph
G = (V, E), and defines the following auxiliary matrices:
1. Incidence matrix A € RV*F: for each edge ¢ = (u,v), the column of A indexed
by e equals either 1, — 1, or 1, — 1,.
2. Cost matrix C' € RF*E: a diagonal matrix with entry C, . equal to the weight
of edge e.

In a transshipment instance, we are also given a demand vector b € RY satisfying
170 = 0.

Consider now the LP formulation for minimum transshipment: min 1C'f : Af = b, and
its dual, maxb’¢ : ||C’*1AT¢HOo < 1. Let us define the solutions to the primal and dual
formulations as flows and potentials:

Definition 7.2.2: Flow

Given a transshipment instance, a flow vector (or flow) is a vector f € RE satisfying
the primal constraints Af = b, and it has cost 1C'f. The flow minimizing 1C'f is called
the optimal flow of the transshipment instance. For any o > 1, an a-approzimate flow
is a flow whose value 1C'f is at most « times the minimum possible (over all flows).

Definition 7.2.3: Potential

Given a transshipment instance, a set of potentials (or potential) is a vector ¢ € RV
satisfying the dual constraints ||C~'AT¢|| < 1. The potential maximizing b”¢ is
called the optimal potential of the transshipment instance.

For convenience, we will treat potentials as functions on V'; that is, we will use the
notation ¢(v) instead of ¢,.

Definition 7.2.4: Flow-potential pair

For any flow f € R¥ and potential ¢ € RY, the pair (f,¢) is called a flow-potential
pair. For a > 1, (f, ¢) is an a-approzimate flow-potential pair if 1Cf < a b’ ¢.

Fact 7.2.5

If (f, ¢) is an a-approximate flow-potential pair, then f is an a-approximate flow.

Proof. Let f* be the optimal flow. The two LPs min1Cf : Af = b and maxb’¢ :
HC‘IAT¢>||OO < 1 are duals of each other, so by (weak) LP duality, the potential ¢ sat-

isfies bT¢p < 1Cf*. Since (f,¢) is an a-approximate flow-potential pair, we have 1C'f <
abley < alCf. O
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Definition 7.2.6: opt

Given a transshipment problem and demand vector b, define opt(b) as the cost of the
optimal flow of that instance, that is:

opt(b) := fgl]icrzlb 1Cf.

When the underlying graph G is ambiguous, we use the notation opt(b) instead.

7.2.3 Parallel Shortest Path Preliminaries

The definitions below are confined to the parallel algorithms in this chapter, so a reader
primarily interested in the sequential transshipment algorithm (Theorem Section
may skip these.

We first introduce a notion of approximate SSSP distances which we call approximate
SSSP potentials.

Definition 7.2.7: Approximate s-SSSP potential

Given a graph G = (V, E) and a source s, a vector ¢ € R is an a-approximate
s-SSSP potential if:

1. Forall v € V, ¢(v) — ¢(s) > L -d(s,v)

2. For each edge (u,v), |¢(u) — ¢(v)| < w(u,v).
When the source s is either irrelevant or clear from context, we may use a-approzimate
SSSP potential (without the s) instead.

Observe that the approximate SSSP potential problem is slightly more stringent than
simply approximate shortest path distances: the second condition of Definition [7.2.7] requires
that distances satisfy a sort of approximate subtractive triangle inequality. To illustrate why
this condition is more restrictive, imagine a graph on three vertices s, u, v, with d(s,u) =
d(s,v) = 100 and d(u,v) = 1, and let o := 10/9. Then, the distance estimates d(s) = 0
and d(u) = 90 and d(v) = 100 are a-approximate SSSP distances with source s, but the
vector ¢ with ¢(s) = 0 and ¢(u) = 90 and ¢(v) = 100 is not a (1 + €)-approximate SSSP
potential because it violates the second condition of Definition for edge (u,v): we have

lp(u) — p(v)| =10 > w(u,v) = 1.

Observation 7.2.8

An a-approximate s-SSSP potential is also an a-approximate potential for the trans-
shipment instance with demands ) (1, — 1) (but the converse is not true).
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Observation 7.2.9

Given a graph G = (V, E) and a source s, any a-approximate s-SSSP potential ¢
satisfies |p(u) — ¢(v)| < d(u,v) for all u,v € V.

Proof. Let u = vg,vy,...,v, = v be the shortest path from s to v. By property (2), we have

L

> " 6(vi) — d(vioy)

=1

|9(u) — p(v)] <

Observation 7.2.10

If ¢ is an a-approximate s-SSSP potential, then ¢ + ¢ - 1 is also one for any scalar
¢ € R. Therefore, we can always assume w.l.o.g. that ¢(s) = 0. In that case, by
property (1), we also have ¢(v) > 0 for all v € V.

Observation 7.2.11

Given two vectors ¢; and ¢, that satisfy property (2), the vectors ¢umin, Pmax € RY

defined as @iy (v) := min{¢;(v), P2(v)} and Pax(v) := max{p;(v), po(v)} forallv € V
also satisfy property (2).

We now generalize the notion of SSSP potential to the case when the “source” is a subset
S C V, not a single vertex. Essentially, the definition is equivalent to contracting all vertices
in S into a single source s, taking an s-SSSP potential, and setting the potential of each
vertex in S to the potential of s.

Definition 7.2.12: Approximate S-SSSP potential

Given a graph G = (V, E) and a vertex subset S C V, a vector ¢ € RY is an a-
approximate S-SSSP potential if:
0. For all s € S, ¢(s) takes the same value

1. Forallv e Vand s € S, ¢(v) — ¢(s) > é -d(s,v)

2. For each edge (u,v), |¢(u) — ¢(v)| < w(u,v).
When the set S is either irrelevant or clear from context, we may use a-approrimate
SSSP potential (without the S) instead.
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Observation 7.2.13

Given a graph G = (V, E) and a vertex subset S C V| let G’ be the graph with all
vertices in S contracted into a single vertex s’. Then, if ¢ is an a-approximate S-SSSP
potential, then the vector ¢’ defined as ¢'(v) = v for v € V' \ S and ¢'(s") = ¢(s) for
some s € S is an a-approximate s-SSSP potential in G.

Also, we will need the notion of a spanner throughout this chapter:

Definition 7.2.14: Spanner

Given a graph G = (V, E') and a parameter « > 1, a subgraph H C G is an a-spanner
of G if for all u,v € V, we have dg(u,v) < dy(u,v) < adg(u,v).

Polynomial Aspect Ratio Throughout this chapter, we assume that the initial input
graph for the approximate SSSP problem has polynomially bounded aspect ratio, defined
below:

Definition 7.2.15: Aspect ratio

maxy ey dg(u,v)
minquEV dG(uav) ’

The aspect ratio of a graph G = (V, F) is the quantity

This assumption can be safely assumed: there is a reduction by Klein and Subrama-
nian [61] (also used by Cohen [28]) that transforms the (1 + €)-approximate SSSP problem
on a graph with arbitrary, nonnegative weights to solving (1 + ¢/2)-approximate SSSP on a
collection of graphs of total size O(mlogn), each with polynomially bounded aspect ratio,
and requiring an additional O(mlogn) work and O(logn) time. Since polynomially bounded
aspect ratio is a common assumption in graph optimization problems, we will not present
this reduction for sake of self-containment.

Since our SSSP algorithm is recursive, and the SSSP problem that we solve is actually
the (slightly more general) SSSP potential problem, we do not apply the reduction of Klein
and Subramanian again in each recursive call. Rather, we take some care to show that the
aspect ratio does not blow up over recursive calls.

For the ¢;-embedding and transshipment problems, we will handle the aspect ratio issue
differently. For the /;-embedding problem, we will explicitly require that the input graph
has aspect ratio at most n® for some fixed constant C' (which can be made arbitrarily
large). In particular, this assumption translates over in our theorem statement for parallel
¢1-embedding (Theorem . For the transshipment problem, we will not assume that the
graph has polynomial aspect ratio, but every time we recursively call transshipment, we will
ensure that the demand vector has small, integral entries in the recursive instance. Assuming
this guarantee on the demand vector, we reduce the transshipment problem to the case when
the graph also has polynomial aspect ratio like in the SSSP case, but here, the reduction is
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simple enough that we include it in this chapter for completeness (Lemma [7.3.5)).

7.3 The Recursive Algorithm

Our algorithm will recursively cycle through three problems: approximate SSSP potentials,
approximate transshipment, and ¢;-embedding. For the /;-embedding and SSSP potential
problems, we will always assume that the input graph has aspect ratio at most n® for
some arbitrarily large but fixed constant C' > 0 (that remains unchanged throughout the
recursion). The transshipment problem will require no bound on aspect ratio: we provide
a simple transformation on the graph to ensure that the aspect ratio is polynomial. Let us
now define the work required to solve the three problems below:

1. Wembea(m) and Tempea(m) are the work and time to ¢1-embed a connected graph with m
edges and aspect ratio at most n° into O(logn) dimensions with distortion O(log'’® n),
where the O(-) hides an arbitrarily large but fixed constant.

2. Wsssp(m, €) and Tsssp(m, €) are the work and time to compute an (1 + €)-approximate
SSSP potential of a connected graph with m edges and aspect ratio at most O(n®),
where the O(-) hides a factor clog®n for an arbitrarily large but fixed constant ¢ > 0.

3. Wrs(m,€) and Trg(m,€) are the work and time to compute a (1 + €)-approximate
transshipment instance of a connected graph with m edges, where the demand vector
b is integral and satisfies |b,| < n — 1 for all vertices v.

The following is the main result of Section [7.3.2}

Theorem 7.3.1: ¢1-embedding given SSSP on smaller instances

Let G = (V, E) be a connected graph with n vertices and m edges with aspect ratio
M, let f > 1 be a parameter, and let A be an algorithm that inputs (i) a connected
graph on at most m/f vertices and edges with aspect ratio O(5%M) and (ii) a source
vertex s, and outputs a (1 + 1/logn)-approximate s-SSSP potential. Then, there
is an algorithm that computes an /;-embedding of G into O(logn) dimensions with
distortion O(3%1log®®n) and calls A at most O(log?n) times in parallel, plus O(m)
additional work and polylog(n) additional time.

Wembea(m) < O(log®n) - Wessp(dm/log* n, 1/logn) + O(m) for any fixed, arbitrarily
small constant § > 0, and Tympea(m) < Tsssp(0m/ log* n, 1/ logn) 4 polylog(n).

Proof. Apply  Theorem m with = %log2 n, obtaining distortion
O(B%10g® n) = O(log"* n). O
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The following is a corollary of our sequential transshipment result in Section [7.4] which
constitutes our main technical contribution of this chapter:

Corollary 7.3.3: Parallel SSSP given ¢;-embedding

Given an undirected graph with nonnegative weights and polynomial aspect ratio, and
given an (1-embedding of the graph with polylog(n) distortion in O(logn) dimensions,
there is a parallel algorithm to compute a (1+¢)-approximate minimum transshipment
instance in O(me2) work and polylog(n)e2 time.

The following is Sherman’s framework for the minimum transshipment problem, for which
we provide a self-contained treatment through the multiplicative weights method in Sec-
tion . This is where the error boosting takes place: given a lossy polylog(n)-approximate
¢1-oblivious routing algorithm encoded by the matrix R, we can boost the error all the way
to (14 €) for transshipment. The only overhead in Sherman’s framework is an additive O(m)
work and polylog(n) time (where these polylogarithmic factors depend on the approximation
of the ¢;-oblivious routing), which is ultimately what makes the recursion work out.

Theorem 7.3.4: Parallel Theorem 7.4.2 with extra log(n/e) factor

Given a transshipment problem, suppose we have already computed a matrix R satis-
fying:
1. For all demand vectors b € R",

opt(b) < 1Rb < k - opt(b) (7.1)

2. Matrix-vector products with R and RT can be computed in M work and
polylog(n) timd]
Then, for any transshipment instance with demand vector b, we can compute a flow
vector f and a vector of potentials ¢ in O(k2(m +n + M) e2) time that satisfies:

L ||Cf]l, < (1 + €T < (1 + €) opt(b)
2. opt(Af —b) < Bopt(d)

®M can potentially be much lower than the number of nonzero entries in the matrix R if it can
be efficiently compressed.

Lastly, there is one minor mismatch: Corollary [7.3.3] assumes that the graph has poly-
nomial aspect ratio, while the problem for Wrg(:) does not assume such a thing, but rather
assumes that the demand vector has entries restricted to {—(n—1), —(n—2),...,n—2,n—1}.
It turns out that given this restriction on the demand vector, the polynomial aspect ratio of
the graph can be obtained for free. We defer this proof to Section [7.10.1]
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Lemma 7.3.5: Aspect ratio guarantee

Given a transshipment instance with graph G' = (V, E) with n vertices and m edges
and an integer demand vector b satisfying |b,| < M for all v € V', we can transform G
into another graph G on n vertices and at most m edges such that G has aspect ratio
at most n*M, and optg(b) < optz(b) < (1 + 1/n?) opty(b). The transformation takes

O(m) work and polylog(n) time.

Corollary 7.3.6

Wis(m, €) < Wembea(m) 4+ O(m/€?). That is, outside of an ¢;-embedding into O(logn)
dimensions with distortion O(log'®®n), the additional work to compute (1 + ¢)-
approximate transshipment is O(m/e?), and the additional time is O(1/€?).

Proof. By assumption, the demand vector b, is integral and satisfies |b,| < n — 1 for all
vertices v. Apply Lemmal[7.3.5with M := n—1 so that the aspect ratio of the modified graph
G is at most n®, which is polynomial, and the optimal solution changes by factor at most
(1+1/n?). Compute an £1-embedding of G into polylog(n) dimensions (in Wemped (m) work),
and then apply Corollary[7.3.3| with approximation factor (1+¢/2). The final approximation
factor is (1 +1/n?)(1+ ¢/2), which is at most (1 +¢) for € > Q(1/n?). (If e = O(1/n?), then
an algorithm running in time O(1/€%) > O(n?) is trivial.) O

We now present the reduction from approximate SSSP to approximate transshipment,
partially inspired by a similar routine in [I4]; for completeness, we give a self-contained proof
of the reduction in Sections and in the form of this theorem:

Theorem 7.3.7

Let G = (V, E) be a graph with n vertices and m edges, and let € > 0 be a parameter.
Given graph G, a source s € V', and an ¢;-embedding of it into O(logn) dimensions
with distortion polylog(n), we can compute a (1 + ¢)-approximate SSSP tree and
potential in additional O(m/e?) work and O(1/€®) time.

Corollary 7.3.8

Wsssp(m, 6) < Wembed(m) + Cj(m/ez) and Tsssp(m, 6) < Tembed(m) + 0(1/62).

Proof. This is essentially Theorem [7.3.7] in recursive form. O

Wsssp(m, €) < O(log®n) - Wsssp (dm/ logZn, 1/logn) + O(m/€?) and Tsggp(m,e) <
Tsssp(0m/log?n, 1/logn) + O(1/€?) for any fixed, arbitrarily small constant § > 0.
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Proof. Follows directly from Corollaries [7.3.2] and [7.3.8] [

Corollary 7.3.10

Wsssp(m, E) S O(m/€2) and Tsssp(m, 6) S 0(1/62).

Proof. Observe that in the recursion of Corollary [7.3.9] by setting § > 0 small enough, the
total graph size O(log?n) - dm/log®n < m/2 drops by at least half on each recursion level.
The time bound follows immediately, and the total work is dominated by the work at the
root of the recursion tree, which is O(m/e?). O

Finally, Theorem follows from Corollaries [7.3.2] and [7.3.10, and Theorem and
Theorem [7.1.2] follow from the addition of Theorem [7.3.7] and Corollary [7.3.6] respectively.

7.3.1 /(;-Embedding from Approximate SSSP Potential

In this section, we briefly overview our ¢;-embedding algorithm, which is necessary for The-
orem [7.3.1] and hence, the reduction from ¢;-embedding to smaller instances of approximate
SSSP potentials. Our ¢;-embedding algorithm is very similar to Bourgain’s embedding as
presented in [74], except utilizing approximate SSSP instead of exact, as well as slightly
simplified at the expense of several logarithmic factors. Due to its similarily, we defer its
proof to Section [7.10]

Theorem 7.3.11

Let G = (V, E) be a graph with n vertices and m edges, and let A be an algorithm
that inputs any vertex set S C V and outputs a (1 + 1/logn)-approximate S-SSSP
potential of G. Then, there is an algorithm that computes an ¢;-embedding of G into
O(logn) dimensions with distortion O(log*®n) and calls A at most O(log®n) times,
plus O(m) additional work and polylog(n) additional time.

We will focus our attention on a slightly different variant which we show implies Theo-

rem [Z.3.1T}
Lemma 7.3.12

Let G = (V, E) be a graph with n vertices and m edges, and let .4 be an algorithm
that inputs any vertex set S C V and outputs a (1 + 1/logn)-approximate S-SSSP
potential of G. Then, there is an algorithm that computes an ¢;-embedding of G into
O(log®n) dimensions with distortion O(log®n) and calls A at most O(log®n) times,
plus O(m) additional work and polylog(n) additional time.

Lemma is proved in Section We now show that Lemma implies
Theorem [7.3.11] Since the ¢; and ¢, metrics are at most a multiplicative vk factor apart

in dimension k, the embedding of Lemma [7.3.12] has distortion O(log®n) - 1/O(log?n) =
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O(log" n) in the ¢, metric. Next, apply Johnson-Lindenstrauss dimensionality reduction [52]
on this set of vectors, reducing the dimension to O(logn) with a constant factor increase in
the distortion. We now move back to the ¢; metric, incurring another O(y/logn) factor in
the distortion, for a total of O(log*® n) distortion.

7.3.2 Sparsification and Recursion to Smaller Instances

In this section, we briefly overview the main ideas behind our sparsification process in order
to reduce the ¢;-embedding problem to approximate SSSP instances of sufficiently smaller
size:

Theorem: Restatement of Theorem 7.3.1

Let G = (V, E) be a connected graph with n vertices and m edges with aspect ratio
M, let B > 1 be a parameter, and let A4 be an algorithm that inputs (i) a connected
graph on at most m/j vertices and edges with aspect ratio O(52M) and (ii) a source
vertex s, and outputs a (1 + 1/logn)-approximate s-SSSP potential. Then, there
is an algorithm that computes an /;-embedding of G into O(logn) dimensions with
distortion O(5%1og®° n) and calls A at most O(log?n) times in parallel, plus O(m)
additional work and polylog(n) additional time.

One key tool we will use is the concept of wultra-sparse spanners, introduced by

Elkin and Neiman [34]. Here, we will rename them to wltra-spanners to further empha-
size their connection to wultra-sparsifiers in [63, 90]. These are spanners that are so sparse
that they are almost “tree-like” when the graph is sparse enough: a graph with (n — 1) +¢
edges for some small ¢ (say, t = m/polylog(n)). We will utilize the following ultra-spanner
construction, which is adapted from the one of [82]; while theirs is not ultra-sparse, we
modify it to be, at the expense of an additional k factor in the stretch. The ultra-spanner
algorithm is deferred to Section

Given a weighted graph G with polynomial aspect ratio and a parameter k > Q(1),

there is an algorithm to compute a k?-spanner of G with (n — 1) + O(™%") edges in

O(m) work and polylog(n) time.

Why are ultra-spanners useful for us? Their key property, stated in the lemma below, is
that we can compute an a-approximate SSSP potential on an ultra-spanner by recursively
calling a-approximate SSSP potentials on a graph with potentially much fewer vertices. To
develop some intuition on why this is possible, observe first that if a connected graph has
(n — 1) edges, then it is a tree, and SSSP is very easy to solve on trees. If the graph has
(n—1)+t edges instead for some small value of ¢, then the graph is almost “tree-like” outside
of at most 2t vertices: take an arbitrary spanning tree, and let these vertices be the endpoints
of the t edges not on the spanning tree. We want to say that the graph is “easy” outside a

124



graph on 2t vertices, so that we can solve a SSSP problem on the “hard” part of size O(t)
and then extend the solution to the rest of the graph in an efficient manner. This is indeed
our approach, and it models closely off the concept of a j-tree by Madry [77], which is also
used in Peng’s recursive max-flow algorithm [90].

This recursion idea can be considered a wertex-sparsification step, following the edge-
sparsification that the ultra-spanner achieves. We package the vertex-sparsification in the
lemma below; while this lemma works for all ¢, the reader should imagine that
t = m/polylog(n), since that is the regime where the lemma will be applied. Due to its
length and technical involvement, the proof is deferred to Section

Lemma 7.3.15

Let G = (V, E) be a connected graph with aspect ratio M with n vertices and (n—1)+t
edges, and let @ > 0 be a parameter. Let A be an algorithm that inputs a connected
graph on at most 70¢ vertices and edges and aspect ratio O(M ) and outputs an a-
approximate s-SSSP potential of that graph. Then, for any subset S C V, we can
compute an a-approximate S-SSSP potential of G through a single call to A, plus
O(m) additional work and polylog(n) additional time.

We now prove Theorem [7.3.1] assuming Lemma
Proof (Theorem . Invoke Lemma with k := Cflogn for a large enough con-
stant C' > 0, producing a spanner H with (n — 1) + O(%) edges and stretch at most
k> = O(B%*log’n). Since H is a spanner, we have min, ey dg(u,v) > min, ey dg(u, v)
and max, yey dg(u,v) < k*max, ey dg(u,v), so H has aspect ratio O(32M). Since G
is connected, we have O(mlogn/k) < m/(708) for C large enough, so H has at most
(n — 1) + m/(7083) edges. Then, apply Lemma on H with t := m/(708), a =
1 + 1/logn, and the algorithm A, producing an algorithm Ay that inputs any vertex set
S C V and outputs an (1 + 1/logn)-approximate S-SSSP potential on H through a single
call to A, plus O(m) additional work and polylog(n) additional time.

Next, apply Theorem on the spanner H with algorithm Ay, embedding H into
O(logn) dimensions with distortion O(log*®n) through O(log?n) calls to Ay, which in turn
makes O(log?n) calls to A; the additional work and time remain O(m) and polylog(n),
respectively.

Finally, since H is a spanner for G with stretch O(3%log®n), the ¢;-embedding of H
with stretch O(log*® n) is automatically an ¢;-embedding of G' with distortion O(5%log®n) -
O(log"® n) = O(3?1og"" n). O

7.4 [(1-Oblivious Routing and Sequential Transshipment

This section is dedicated to the sequential transshipment result (Theorem restated
below) and constitutes our main technical contribution of this chapter.
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Theorem: Restatement of Theorem 7.1.4

There is an algorithm that, given an undirected graph with nonnegative weights and
polynomial aspect ratio, computes a (1+ €)-approximation to minimum transshipment
in time O((mlog' n 4+ nlog" n) =2 (loglogn)°M).

Throughout the section, we make no references to parallel algorithms, keeping all our
algorithms entirely sequential in an effort to focus solely on Theorem [7.1.4, Nevertheless,
to a reader with parallel algorithms in mind, it should be clear that all algorithms in this
section can be parallelized to require polylog(n) parallel time. To streamline the transition
to parallel algorithms in the rest of this chapter, we package a parallel version of the main
routine in this section in an easy-to-use statement, Corollary

7.4.1 Improved /;-Oblivious Routing: Our Techniques

The key technical ingredient in our transshipment algorithm is an improved ¢;-oblivious
routing, scheme. Our algorithm begins similarly to Sherman’s [97]: compute an ¢;-embedding
into low dimensions at a small loss in approximation. Sherman chooses dimension O(+/logn)
and loses a 20VIoen) factor in the distortion, and then constructs an oblivious routing in the
embedded space in time exponential in the dimension. Our oblivious routing is polynomial in
the dimension, so we can afford to choose dimension O(logn), giving us polylog(n) distortion.
The benefit in the ¢;-embedding is that we now have a nice geometric property of the vertices,
which are now points in O(logn)-dimensional space under the ¢; metric.

At this point, let us provide some intuition for the oblivious routing problem in ¢; space.
Suppose for simplicity that the dimension is 1 (i.e., we are on the real line) and that all
vertices have integer coordinates. That is, every vertex v € V' is now an integer on the real
line, i.e., V C Z. We will now (informally) define the problem of oblivious routing on the
linef

1. Our input is a set of points V' C Z. There is also a function b : V' — R of demands

with Y~ o b(v) = 0 that is unknown to us.

2. On each step, we can choose any two points z,y € Z and a scalar ¢ € R, and “shift”
¢ times the demand at x to location y. That is, we simultaneously update b(z) <+
b(x) —c-b(z) and b(y) < b(y) +c-b(x). We pay c¢-b(z) - | — y| total cost for this step.
Again, we do not know how much we pay. Let an iteration be defined as one or more
such steps executed in parallel.

3. After a number of iterations, we declare that we are done. At this point, we must be
certain that the demand is 0 everywhere: b(z) = 0 for all = € Z.

4. Once we are done, we learn the set of initial demands, sum up our total cost, and

30ur formal definition of oblivious routing is in matrix notation, and is considerably less intuitive. There-
fore, we hope to present enough of our intuition in this section.
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Figure 7.2: Oblivious routing in 1-dimensional (¢;-) space. Here, there are only two locations
with nonzero demand at the beginning: 4+1 demand at point 5 and —1 demand at point 14.
The optimal routing for each b; has cost 18, and the routing costs of iterations t = 1,2,3,4,5
are 1, 3, 5, 3, and 9, respectively.

compare it to the optimal strategy we could have taken if we had known the demands
beforehand. We would like our cost to be comparable with this retrospective optimum.
In particular, we would like to pay at most polylog(n) times this optimum.

We maintain functions by, by, by ... : Z — R that track how much demand remains at
each (integer) point after each iteration. Given a demand vector (function) b: V' — R, every
vertex v € V has an initial demand by(v) := b(v), and these demands sum to 0. Consider
the following oblivious routing algorithm: for each iteration ¢t = 1,2,..., every point x € Z
with z = 27! mod 2! sends b;(z)/2 flow to point z — 2! and b;(x)/2 flow to point x + 2¢71;
let byy1(z) be the new set of demands (see Figure [7.2)).

This is actually Sherman’s oblivious routing in 1-dimensional space. He proves the fol-
lowing two properties of the routing:

1. After each iteration ¢, the optimal routing for the remaining points can never increase.

(In Figure , the optimal routing of each b, is exactly 9.)

2. The routing cost at each iteration ¢ is at most the optimal cost of routing b;. (In
Figure the routing costs of iterations ¢t = 1,2,3,4 are 1, 3, 5, and 3, respectively.)

Let us assume that V' C [0,1,2,...,n¢ for some constanct ¢, that is, all points in V'
are nonnegative, polynomial-sized integers. Then, after [log,(n®)] = O(logn) iterations, all
points are either on 0 or 2M°€2(")1 " Thus, moving all demand from 0 to 2M°&2(")1 finishes the
oblivious routing. From the two properties above, this oblivious routing can be shown to be
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O(log n)-competitive.

We believe this simple scheme provides a good intuition of what an oblivious routing
algorithm requires. In particular, it must be unbiased, in that demand from a given vertex
must be spread evenly to the left and right. This is because we do not know where the
demands lie, so our best bet is to spread equal amounts of demand left and right.

Sherman’s oblivious routing extends this idea to higher dimensions. The actual rout-
ing is more complicated to describe, but as an example, on iteration ¢ = 1, a point x =
(1,1,1,...,1) will need to send b(z)/2* flow to each of the 2% points in {0,2}*. In other

words, the running time can be exponential in the dimension.

This is where our oblivious routing algorithm deviates from Sherman’s. To avoid the
issue of sending flow to too many other points, we make use of random sampling: on each
iteration, every point sends its flow to polylog(n) randomly chosen points close-by. These
random points need to be correlated sufficiently well so that we can control the total number
of points. (In particular, we do not want the number of points to increase by factor O(logn)
each iteration, which would happen on a naive attempt.)

To solve this issue, we use the concept of randomly shifted grids popular in
low-dimensional computational geometric algorithms [49]: overlay a randomly shifted grid of
a specified size W in the R*-dimensional space. Every point sends a fraction of its demand
to (say) the midpoint of the grid cell containing it.ﬁ The benefit in grid shifting is that
many nearby points can coalesce to the same midpoints of a grid, controlling the growth of
the number of points. We compute s = polylog(n) such grids, with each point sending 1/s
fraction to the midpoint of each grid; this is to control the variance, so that we can apply
concentration bounds to show that we are still approximately unbiased from each point.

7.4.2 Sherman’s Framework

Below, we state a paraphrased version of Sherman’s framework [97]. For the simplest refer-
ence, see Corollary 1 and Lemma 4 of [60]. We also provide a proof via multiplicative weights
in Section [7.7, whose running time suffers an additional factor of log(n/e) due to a binary
search overhead. For Theorem we will use the theorem below, while for the parallel
algorithms, the weaker Theorem suffices.

4For notational simplicity, our algorithm will actually send to the “lower-left” corner of each grid, but for
this section, midpoint is more intuitive to think about.
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Theorem 7.4.2: Sherman, paraphrased

Given a transshipment problem, suppose we have already computed a matrix R satis-
fying:

1. For all demand vectors b € R™, opt(b) < 1Rb < k - opt(b)

2. Matrix-vector products with R and RT can be computed in M time

Then, for any transshipment instance with demand vector b, we can compute a flow
vector f and a vector of potentials ¢ in O(k*(m + n + M)log(m)(e~? + log(1/3)))
sequential time that satisfies:

L ||Cf]l, € (1 +€)bTd < (1 +¢€) opt(b)
2. opt(Af — b) < Bopt(h)

The matrix R encodes the oblivious routing algorithm. Also, intuitively, the more efficient
the oblivious routing, the sparser the matrix R, although this relation is not as well-defined.
Nevertheless, there is an equivalence between oblivious routing schemes and matrices R
that satisfy requirement (1) of Theorem [7.4.2] But since Sherman’s framework uses steep-
est descent methods that involve matrix algebra, a matrix R with efficient matrix-vector
multiplications is most convenient for the framework.

Our main technical result is computing such a matrix R efficiently:

Theorem 7.4.3: Computing R

Given a transshipment problem, we can compute a matrix R with
O(nlog® n(loglog n)°M) nonzero entries, such that for any demand vector b,

opt(b) < 1Rb < O(log*® n) - opt(b).

The algorithm succeeds w.h.p., and runs in O(mlog®n + nlog™ n(loglogn)®™) se-
quential time.

With this fast routing algorithm in hand, our main theorem, Theorem follows
immediately. Our proof uses low-stretch spanning trees [4], so for a self-contained rendition,
we remark after the proof that low-stretch spanning trees can be removed at the expense of
another logn factor.

Proof of Theorem[7.1.4]. Apply Theorem with the parameters x := O(log"®n) and
M = O(nlog’n(loglogn)°®)  guaranteed by Theorem m along with
B = 0O(e/(lognloglogn)). This takes time

O(log’ n-(m+nlog® n)-logn-e2-(loglog n)°Y) = O((mlog' n+nlog" n)e %(loglog n)°W),

and outputs a flow f with 1C'f < (1 + ¢) opt(b) and opt(Af — b) < Bopt(b).
To route the remaining demand Af — b, for O(logn) independent iterations, compute a
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low-stretch ~ spanning tree in O(nlognloglogn) time with expected stretch
O(lognloglogn) [4] and solve (exact) transshipment in linear time on the tree. In each
iteration, the expected cost is at most O(lognloglogn) - 5 opt(b) = eopt(b) for an appropri-
ate choice of 3, so w.h.p., one iteration has cost at most twice the expectation. Let f’ be
this flow, which satisfies 1C'f’ < 2eopt(b) and Af’ = Af —b. The composed flow f — f' is
our final flow, which satisfies 1C(f — f') < 1Cf +1Cf < (14 3€) opt(b) and A(f — f') = b.
Finally, to obtain a (1 4 €)-approximation, we can simply reset € < €/3. O]

Remark 7.4.4

To eliminate the use of low-stretch spanning trees, we can set 5 := €/n instead, picking
up another logn factor in Theorem [7.4.2] Then, we can route the remaining demand
along a minimum spanning tree, which is an (n — 1)-approximation of optimum, or at
most (n — 1) fopt(b) < eopt(b).

7.4.3 Polynomial Aspect Ratio

Throughout this section, we assume that the input graph has polynomial aspect ratio, since
that is assumed in Theorem [7.1.4

7.4.4 Reduction to ¢/; Metric

The reduction to the ¢; metric is standard, via Bourgain’s embedding;:

Definition 7.4.5

For p > 1, an {,-embedding of a graph G = (V, E) with distortion o and dimension k
is a collection of vectors {z, € R* : v € V} such that

Vu,v € Vi dg(u,v) < ||zy — 20|, < ada(u,v).

Theorem 7.4.6: Fast Bourgain’s embedding

Given a graph with m edges, there is a randomized O(mlog®n) time algorithm that
computes an f;-embedding of the graph with distortion O(log’®n) and dimension
O(logn).

Proof (Sketch). Apply the fast embedding algorithm of [74] in ¢, which runs in O(mlog®n)
randomized time and w.h.p., computes an ¢»-embedding of the graph with distortion O(logn)
and dimension O(log®n). Next, apply Johnson-Lindenstrauss dimensionality reduction [52]
on this set of vectors, reducing the dimension to O(logn) with a constant factor increase in
the distortion. Lastly, since the ¢; and ¢, metrics are at most a multiplicative vk factor apart
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in dimension &, this same set of vectors in O(logn) dimensions has distortion O(log"® n) in
the ¢; metric. 0

Finally, since our input graph is assumed to have polynomial aspect ratio, so do the em-
bedded points under the ¢; metric. In particular, suppose that before applying Theorem|[7.4.6
we scaled the graph G so that the smallest edge had length 1. Then, the embedding satisfies
the following;:

Assumption 7.4.7: Polynomial aspect ratio

For some constant ¢ > 0, the vectors {z, : v € V} satisfy 1 < d(u,v) < n for all
u,v € V.

7.4.5 Oblivious Routing on ¢; Metric

In this section, we work under the ¢; metric in O(logn) dimensions (the setting established
by Theorem [7.4.6|) with the additional Assumption Our main technical result is:

Theorem 7.4.8

We can compute a matrix R with O(nlog® n(loglogn)°®") nonzero entries, such that
for any demand vector b,

opt(b) < 1Rb < O(log®n) - opt(b).

The algorithm succeeds w.h.p., and runs in O(nlog' (loglogn)°™M) sequential time.

Together with the O(log'® n) additional distortion from Theorem , this proves The-
orem

Before we begin with the algorithm, we first make a reduction from “w.h.p., for all b” to
the weaker statement “for each b, w.h.p.”. The former requires that w.h.p., the statement
holds for every demand vector b, while the latter requires that for any given demand vector
b, the statement holds w.h.p. (Since there are uncountably many such b, the latter does not
imply the former in general.) This simplifies our argument, since we only need to focus on a
given demand vector b, which will often be fixed throughout a section. Before we state and
prove the reduction, for each v € V', let us define x, : V' — R as the function that is 1 at v
and 0 elsewhere.

Lemma 7.4.9

Suppose a randomized algorithm outputs a matrix R such that for any given demand
vector b, we have opt(b) < 1Rb with probability 1, and 1Rb < k - opt(b) w.h.p. Then,
this same matrix R satisfies the following stronger property: w.h.p., for any demand
vector b, we have opt(b) < 1Rb < & - opt(b).

131



Proof. W.h.p., the matrix R satisfies 1R(xy, — Xv) < & - 0pt(xu — Xo) for each of the O(n?)
demand vectors x, — X (u,v € V). We claim that in this case, R actually satisfies opt(b) <
1Rb < k - opt(b) for all demand vectors b.

Fix any demand vector b, and suppose that the flow achieving opt(b) routes f(u,v) > 0
flow from u to v for each u,v € R¥, so that b = > f(u,v) - (xu — xv) and opt(b) =
> uw [ (1, 0) - Tu —v. Then, we still have opt(b) < 1Rb by assumption, and for the other
direction, we have

1Rb=1R- Z f(U, U)(Xu - Xv) < Z f(u, U>1R(Xu - XU)

u,v

<k fu,v)opt(xu — xu) = # - opt(b),

as desired. ]

We also introduce a specific formulation of a routing that helps in the analysis of our
algorithm:

Definition 7.4.10: Routing

Given a metric space (V,d), a routing is a function R : V x V — R such that
Yu,v € Vi R(u,v) = —R(v,u).
A routing R satisfies demand vector b € RV if

YoeV: ZR(u,v)zbv.

ueV

A routing R has cost
cost(R) := »_ |R(u,v)| - lu—w,
u,veV

and is optimal for demand vector b if it minimizes cost(R) over all routings R’ satisfying
b.

For example, if b = x, — x, for some u,v € V, then one feasible routing (in fact, the
optimal one) is R(u,v) =1, R(v,u) = —1, and R(x,y) = 0 for all other pairs (x,y), which
has cost 21u — v.

Note that cost(R) is actually twice the value of the actual transshipment cost in the ¢,
metric. However, since this notion of routing is only relevant in our analysis, and we are
suffering a polylog(n) approximation anyway, we keep it this way for future simplicity.
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Observation 7.4.11

Given a metric space (V, d) and demand vector b € RV, opt(b) is equal to the minimum
value of 1cost(R) over all routings R that satisfy demand vector b.

We first introduce our algorithm in pseudocode below, along with the following notations.
For real numbers x and W > 0, define |z ]w := |x/W |- W as the greatest (integer) multiple
of W less than or equal to W (so that if W = 1, then |z]w = |z]), and similarly, define
[z]w = [z/W] - W as the smallest (integer) multiple of W greater than or equal to W.

The lines marked tmaginary are actually not executed by the algorithm. They are present
to define the “imaginary” routings R;, which exist only for our analysis. We could have
defined the R} separately from the algorithm, but we decided that including them alongside
the algorithm is more concise and (more importantly) illustrative.

Lastly, we remark that the algorithm does not require the input b € R to be a demand
vector. This observation is important when building the matrix R, where we will evaluate
the algorithm on only the vectors y, for v € V| which are not demand vectors.

Proof Outline. The purpose of the “imaginary” routing R} is to upper bound our actual
cost. For R; to be a reasonable upper bound, it should not increase too much over the
iterations. These properties are captured in the two lemmas below.

Lemma 7.4.12

The total cost of routing on each iteration ¢ (lines|17|and is at most kw - cost(Ry).

Lemma 7.4.13

With probability 1 —n=“®), cost(R; ;) < (1 + =) cost(R}) for each iteration ¢.

logn

The last routing on lines [24) and [25] is handled by the following lemma.

The total cost of routing on lines [24] and [25|is at most O(kw) - cost(R7., ;).

The three lemmas above imply the following corollary:

Corollary 7.4.15: Cost of routing

With probability 1 — n=“(), the total cost of routing in the algorithm is at most
O(kwT') - opt(b).

133



Algorithm 9 Routing(V,b)

Input:
(1) V, a set of n vectors in R* satisfying Assumption [7.4.7, where k = O(logn)
(2) b € RV, a (not necessarily demand) vector

w oy =

>

10:
11:
12:
13:
14:
15:
16:
17:
18:
19:

20:
21:

22:
23:
24:

25:

Initialize w < [logn], s < [log* nloglogn], T + [log, (n°)]
Initialize V) < V
Initialize function by : RY — R satisfying by(v) = b, for all v € V and b(x) = 0 for all
x ¢V
Initialize function R : RY x RY — R as the zero function (i.e., R(x,y) = 0 for all
r,y € RY)
Initialize R : RV x RV — R as the optimal routing satisfying by > Imaginary
for iteration t =0,1,2,...,7T do
W <« w?, a positive integer
Initialize Vi, < 0
Initialize b4, : RV — R as the zero function
Initialize Ry, : RY x RV — R as the zero function > Imaginary
for independent trial j =1,2,...,s do
Choose independent, uniformly random real numbers r1,...,7r, € [0, W)
Define h; : RF — R* as (h;(z)); = |z; + r]w for all i € [k]

for z € V, : by(z) # 0 do
y < h;(z)
Vier < Vi U{y} > Vi is the set of points with flow after iteration ¢
R(z,y) < R(z,y) + bi(x)/s > Send b;(z)/s flow from x to y

R(y,z) < R(y,x) = bi(z)/s
b1 (y) < i1 (y) + be(x) /s
for (z,y) € R* x R*: R¥(z,y) # 0 do > Imaginary
R (hs (), h(9))  Bia(s(2), hi(y)) + i (2,5)/5 > Imaginary: move 1/s
fraction flow
Let y € Vi1 be arbitrary
for x € Vpyy i brya(x) #0 do
R(z,y) + R(z,y) + bri1(xz) > Route all demand in Vg to arbitrary vertex y in

Vi
R(y,z) < R(y,x) — by (x)
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Proof. By applying Lemma [7.4.13| inductively over all ¢, with probability 1 — n=<(1)

1 t 1 t O(logn)
cost(R;, ;) < <1 + @) cost(Ry) = (1 + @) opt(b) < (1 + 10gn) opt(b)

= 0(1) - opt(d).

By Lemmal7.4.12] the cost of routing on iteration ¢ is at most kw - cost(R;) < O(kw)-opt(b).
Summing over all ¢, we obtain a total cost of O(kwT) - opt(b) over iterations 0 through
T. Finally, by Lemma [7.4.14] the cost of routing on lines 24] and [25] is at most O(kw) -
cost(R} ;) < O(kw) - opt(b) as well. O

At the same time, the routing should be “sparse”, to allow for a near-linear time algorithm.
Our sparsity is captured by the following lemma.

For each y,, if we run Algorithm |§| on demands y,, every function b; has O(s) nonzero
values in expectation. Moreover, each function b; can be computed in O(s?) expected
time.

This sparsity guarantee ensures that the matrix R that we compute is also sparse, spec-
ified in the lemma below.

Lemma 7.4.17

We can compute a matrix R such that 1Rb approximates the cost of routing
in Algorithm [9] to factor O(1), and R has O(sTn) = O(nlog® n(loglogn)°M)
nonzero entries. The algorithm succeeds w.h.p., and runs in time O(s*Tnlogn) =
O(nlog' n(loglogn)°W).

Finally, with Corollary and Lemmas [7.4.16] and [7.4.17], we prove Theorem
below:

Proof of Theorem[7.4.8 By Lemma we can compute a matrix R that approximates
the cost of routing in Algorithm |§| to factor O(1). By Corollary this cost of routing is
at most O(kwT) -opt(b), and it is clearly at least opt(b). Thus, R approximates opt(b) by an
O(kwT) = O(log® n) factor. The requirements on R are guaranteed by Lemma . O

Proof of Approximation (Lemmas [7.4.12| and [7.4.13)). We first begin with a few

invariants of Algorithm [9] whose proofs are trivial by inspection:

Invariant 7.4.18

At the end of iteration ¢, R satisfies demand vector ;1.

Proof. Suppose by induction on t that R satisfies demand vector b; at the beginning of
iteration ¢. Recall that for R to satisfy b;,1 at the end of iteration ¢, we must have
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> . R(u,v) = bq(v) for all v by then. For each v, we track the change in ) R(u,v)
and show that the total change on iteration ¢ is exactly byy1(v) — bi(v), which is sufficient
for our claim. By lines |17 and [18] for each x satisfying b;(z) # 0 and each j € [s], the value
>, R(u, hj(z)) increases by b;(z)/s and the value > R(u,x) decreases by b,(x)/s. For each
x with b;(x) # 0, the s decreases add up to a total of b;(z). As for by1(v) —be(v), a demand
of b;(v) is not transferred over to by, (v) if by(v) # 0, and by 1(v) increases by b;(x)/s for each
x,j with h;(z) = v. Altogether, the differences in ) R(u,v) and by41(v) — by (v) match. O

Ry, satisfies demand vector by ;.

Proof. Suppose by induction on ¢ that R; satisfies demand vector b;; the base case t = 0
is trivial. For each v satisfying b:(v) # 0 and each j € [s], the value > Ry, (u,h;(v))
increases by > Rj(z,v)/s (line , which by induction is exactly b;(v)/s. This matches
the increase of by1(h;(v)) by b(v)/s on line [L9] O

Invariant 7.4.20

For each pair (x,y) with Rf, ,(z,y) # 0, z —y has all coordinates an (integral) multiple
of w'.

Proof. The only changes to R;,, are the Ry, (h;(x), h;(y)) changes on line 21} By definition
of h;, we have that h;(u) — h;(v) is a multiple of W = w' for all u, v. O

Lemma: Restatement of Lemma 7.4.12

The total cost of routing on each iteration ¢ (lines 17| and is at most kw - cost(Ry).

Proof. For each trial j € [s], by construction of h;(x) (line[13)), we have 1(h;(z)); — z; < kW,
which incurs a cost of at most |b;(z)/s| - kW in the routing R (lines [I7 and [18). Over all s
iterations, each z € R* with b;(x) # 0 is responsible for at most |b;(x)| - kW cost.

We now bound ) |b,(z)| - kW in terms of cost(R;). By Invariant [7.4.19, for each x with
bt('x) 7& 07
IR @y = [ Y Ri(z.y)| = [bil()]-
Yy Yy

(Here, the summation is over the finitely many y that produce a nonzero summand.) Sum-
ming over all such z, we get

Yo @< Yo Y IRi(@y)l < Y IR (@)l (7.2)

x:be (x)#0 z:be(x2)#0 Y
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By Invariant [7.4.20, we have 1z —y > w'™! for each (z,y) with R} (z,y) # 0. Therefore,

cost(R}) = S R; (e p)| - 1w —y = 3 R ()] - w' . (7.3)
T,y

x?y

Thus, the cost is at most

(7.2) (7-3)
> fb(x)| - kW 2 > IRy (@, y)| - kW = kw - > |R; (2, y)| - w'! 2 kw - cost(RY). (7.4)
x x,Y Y

]

For each t € [T + 1], R} has support size n®").

Proof. For each t € [0,T], by lines [20| and every (z,y) with R;(x,y) is responsible for
creating at most s < O(log” n) nonzero values in R, . Also, R} is supported in V, so it has
support size n®1). Therefore, R} has support size at most

nO(l) . 8T+1 — nO(l) . (O<10g5 n))O(logn/ loglogn) _ nO(l).

Lemma 7.4.23

Fix two points (x,y) with Ry, (x,y) # 0, and fix a coordinate ¢ € [k]. With probability
1 —nM, we have

é; |(hj(2))i = (hi(y))i] < (1 + @) |z — i (7.5)

Proof. Define 0; := x; — y;. First, if §; = 0 <= x; = y;, then (h;(z)); = (h;(y)); with
probability 1, so both sides of are zero.

Assume now that d; > 0. Throughout the proof, we recommend the reader assume
W = 1 so that |z|w is simply |z], etc., since the proof is unchanged upon scaling W.
Define {z}w := = — |z]w, the “remainder” of z when divided by W.

Observe that for each of the s independent trials, (h;(z)); — (h;(y)); = |0; |w with proba-
bility 1 —{6; }w /W and (h;(z)); — (hj(y)); = [6;|w with probability {d;}w /W. In particular,
E[(h;(2))i = (hy(y))i] = s

For j € [s], define random variable X; as the value of ((h;(x)); — (h;(y)); — [6:i]w)/W
on the j’th independent trial, so that X; € {0,1} and E[X;] = {6;}w /W for all j. We can
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express the LHS of (7.5)) as

=105 = ()l = = 3 ()i = (s(w))

= % ZXj + (6 — {di}w). (7.6)

Define p:= 3, E[X;] = (s/W) {d;}w. By Invariant [7.4.20| applied to iteration ¢t — 1, we
know that d; is a multiple of w'~! = W/w, so {§;} > W/w, which means y > s/w. Applying
Chernoff bounds on the variables X, ..., X, € [0,1] with € := 1/logn, we obtain

Pr (DX, = (14 p| < exp(—u/3) < exp (——) — exp(—w(logn)) = 0.
o 3w log®n
This means that with probability 1 — n“(),
1 S ) W s
=3 gl B ST+ (5 (o)
=1 j=1
w
< ?(1 +e)p+ (i — {0ifw)
= (1 +e){ditw + 6 — {di}w
< (14 €)o;
1
= (1+ )|$z’—yi|,
logn
completing (7.5).
Finally, for the case d; < 0, we can simply swap = and y and use the §; > 0 case. O

Proof of Lemma[7.4.13. By lines[20)and 21} every (z,y) with R;(z,y) # 0 is responsible for
a total cost of

S I 3 ) — ) = DS S ) —

j=1 i=1
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We now take a union bound over all such (z,y) (at most n°®) many by Claim [7.4.22)). By
Lemma [7.4.23] we have that with probability 1 — n=“(), the total cost is at most

|Rf($ay)|zs:zk: 1+ 1 i — i) = [ 1+ L IR} (x,y)| - 1o —y
s logn ‘ ! logn o '

j=1 i=1

Summing over all such (z,y), we obtain cost(R;, ;) < (1 + @) cost(Ry), as desired. O

Proof of Sparsity (Lemma [7.4.16]). For the proof of Lemma [7.4.16| we first introduce
the concept of a history graph that tracks the routed flow.

Definition 7.4.24

Define the history graph H to be the following digraph on vertex set V(H) := (Vj X
{oHu(Vix{1HU---U(Vrp1 x{T+1}). Forevery t € {0,1,...,T} and every x,y such
that line [17]is executed at least once on R(x,y), add a directed edge ((z,t), (y,t+ 1))
in H. (By Invariant below, every such z,y must satisfy x € V; and y € V;41.)
A vertex (x,t) € V(H) originates from vertex v € V = V} if there is a directed path
in H from (v,0) to (z,1).

Invariant 7.4.25

For each = with by, 1(x) # 0, we have x € V4.

Proof. Every x € V with value b1 (x) modified in line [19is added into Vi1 in line[17, O

Invariant 7.4.26

For each point v € V and point x € V; where (x,t) originates from v,

Vi € [k] : Ogvi—xiSij.

Proof. We prove the statement by induction on t; the base case t = 0 is trivial. For iteration
t, for each v,x where (z,t) originates from v, we have z; — W < (h;(z)); < x; for all
i € [k], j € [s] by definition of h;. By induction, 0 < v; — x; < 22;11 w’ for all ¢ € [k].
Therefore, the points h;(z) € Vi1, which also originate from v, satisfy

t—1 t

v; — (hj(z));i > v —2; >0 and vi—(hj(x))iSvi—mi—i-WSij—l—W:ij
j=1 j=1

for all ¢ € [k], completing the induction. O
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For each point v € V and iteration ¢t € [T + 1], the expected number of vertices
(x,t) € V(H) that originate from v is O(s).

Proof. Fix an iteration ¢t € [T +1]. Let r := 22;11 w! < 2w'™1; by Invariant [7.4.26) all points

x € V; such that (z,t) originates from v are within the box B := [v; — r,v1] X [vg — 1,19 X
X g — 1 U]

For each trial j € [s]|, consider the set S := {h;(z) : + € B}; note that every y in
lines |17 and [18| for this trial satisfies y € S. We claim that this set has expected size O(1).
To see why, observe that for each ¢ € [k], the value (h;(z)); over all x € B takes two distinct
values with probability /W and one value with probability 1 — /W, and these events are
independent over all 4. Moreover, if ¥’ < k of them take two distinct values, then |S| < 2¥',
and this happens with probability (]f,) (=) (1 — £)**. Overall, the expected size of || is
at most

S ()G () = e (7)) = ()

th—l k 9 O(logn)
<1 =14 ——= =0(1).
() () -ow

Over all s independent trials, the sets S together capture all points y such that (y, t) originates
from v. The expected number of such points (y,t) is therefore at most O(s). O

Proof of Last Routing (Lemma [7.4.14)).

Proof. We can follow the proof of Lemma to obtain (7.4)), where W := w?™! in this
case. By Invariant for each point v € V' and point = € V1 where (z,t) originates
from v, we have Tv —x < kw Z]T:l w’ = O(kw™™). By Assumption [7.4.7, the vertices
v € V are at most n¢ < w’ apart from each other in ¢; distance. This means that the
points x € Vpy, are at most O(kw?™!) apart in ¢; distance. Therefore, the routing on

lines [24] and [25| has cost C' < Y |bri1(z)] - O(kw' ™). Combining this with (7.4)) gives

kweost(R*) > Y by (z)] - kw'™ > Q(C),
which means C' < O(kw) - cost(R} ), as desired. O

Computing the Matrix R. First, we can modify Algorithm [J] to construct the graph
H without changing the running time, since every edge added to H can be charged to one
execution of line [I7

Now for any vector b € RV not necessarily satisfying 1 -b = 0, let R, be the value of R
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once Algorithm 9 is run on b. First, we will henceforth assume the following for simplicity:
Assumption 7.4.28

For each b, every (z,y) is updated at most once in Ry(z,y) throughout Algorithm [9]

Intuitively, Assumption is true with probability 1 because two different randomly
shifted grids in Algorithm [9] align perfectly with probability 0. More specifically, the prob-
ability that h;(z) = hj(2’) for two distinct x,j and 2/, 5" (possibly not even at the same
iteration) is 0.

Lemma 7.4.29

Assuming Assumption [7.4.28| we have that w.h.p., for each b and iteration ¢,

}lkwt S @< Y |Rb(a:,y)].]1x—y§2kwt S @l @)

x:b¢ (x)#0 x,y: by (z)#0 x:b¢(x)#0

Proof. Fix some x € V;, and fix a coordinate i € [k]. For each trial j € [s], the difference
x; — (h;j(x)); is a uniformly random number in [0, W) (where W := w" as before). Define
random variable X; := (z; — (h;(2));)/W € [0,1], and p := >, E[X;] = s/2. Applying
Chernoff bounds on the variables X; with € := 1/4, we obtain

Pr [ X - M‘ > 6#] < exp(—€*/3) < exp (—Q(s)) = n~=W.
j=1
w(1)

Therefore, with probability 1 — n=«",

S (o (y(a))) = ZWX e EW, %w] |

J=1

Summing over all ¢ € [k], we obtain

S e ya) = 3 (i — (y(a))) € EksW, %ksW} |

=1 i=1

At this point, let us assume that every statement holds in the proof so far, which is true w.h.p.
Fix a demand vector b; by Assumption each term in the sum >° . )0 [B(2,y)| -
Iz — y appears exactly once in line [I7] so it must appear on iteration ¢. In particular, the
terms can be exactly partitioned by z. Every z with b;(x) # 0 contributes >, [b;(x)/s] -
1z — h;(z) to the sum (line , which is within [1kW|[b,(2)|, 2kW |b,(2)|]. Summing over
all x proves . O

Therefore, by Lemma [7.4.29] to estimate the final routing cost >, |Ry(z,y)|- 1z —y by
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an O(1) factor, it suffices to compute the value

D kw' > bi(x)]. (7.8)

2:b (x)#0

Remark 7.4.30

The purpose of reducing to summing over the values |b;(x)| is to save a factor s in the
running time; if we did not care about extra polylog(n) factors, we could do without
it.

Assuming Assumption [7.4.28] our goal is to construct a sparse matrix R so that 1Rb
equals . To do so, our goal is to have each coordinate in Rb represent kw'b;(x) for some
t,x with b(x) # 0. This has the benefit of generalizing to general demands b by the following
linearity property:

Every value by(z) for t € {0,1,2,...,T + 1}, x € R¥ is a linear function in the entries
of be RY.

Proof. We show this by induction on ¢; the base case t = 0 is trivial. For each ¢ > 0, the
initialization b;,; as the zero function is linear in b, and by line each update of some
biy1(y) adds a scalar multiple of some b,(z) to byy1(y). Since by 1(y) was linear in b before
the operation, and since b;(x) is linear in b by induction, b;;;(y) remains linear in b. O

To exploit linearity, we consider the set of “basis” functions R, where b = x, for some
v € V. (Again, note that x, is not a demand vector, but we do not require that property
here.)
Proof of Lemma[74.16. We first show by induction on ¢ that if b;(x) # 0 for z € R*, then
(x,t) originates from v; the base case t = 0 is trivial. For each ¢ > 0, the only way some
biy1(y) is updated (line is if there exist © € R¥ with b,(z) # 0 and y = h;(z) for some
J € [s]. By induction, = originates from v, and by definition of the history graph H, there
is a directed edge ((x,t),(y,t + 1)) in H added when line [17]is executed for this pair z,y.
Therefore, there is a path from (v,0) to (y,1) in H, and y also originates from v.

Therefore, for each ¢, the number of points z € R¥ satisfying b,(x) is at most the number
of vertices (z,t) € V(H) originating from v, which by Lemma is O(s) in expectation.

Finally, the functions b, can be computed by simply running Algorithm @ O(s) time is
spent for each (z,t) with b;(z) # 0 (assuming the entries of R, are stored in a hash table),
giving O(s?) expected time for each iteration ¢. O

Proof of Lemma[7.4.17. We run Algorithm [J]for each demand y, over the same randomness
(in particular, the same choices of h;); define by to be the function b; on input x,. Let b
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be the functions on input b. By linearity (Claim [7.4.31)), we have that for each t, z,

= b(v) - b (w (7.9)

veV

By Lemma |7.4.16, the functions by for all ¢, x, can be computed in O(s*Tn) total time in
expectation.

We now construct matrix R as follows: for each ¢,z with b (x) # 0 for at least one .,
we add a row to R with value kw'b}” at each entry v € V. The dot product of this row with

b, which becomes a coordinate entry in Rb, is exactly

S kb (@) - (o) E ku'by(a).

veV

Hence, 1Rb is exactly (7.8)), which approximates the routing cost to factor O(1) by
Lemma [7.4.29} assuming Assumption [7.4.28| (which holds with probability 1). Finally, by
Lemma [7.4.16] R has O(sT'n) entries in expectation.

Lastly, we address the issue that the algorithm only runs quickly in expectation, not
w.h.p. Our solution is standard: run the algorithm O(logn) times, terminating it early
each time if the running time exceeds twice the expectation. Over O(logn) tries, one will
finish successfully w.h.p., so the final running time has an extra factor of O(logn), hence
O(s*Tnlogn). O

7.4.6 Parallel Transshipment

By inspection, the entire Algorithm |§| is parallelizable in O(m) work and polylog(n) time.
The only obstacle to the entire ¢;-oblivious routing algorithm is the initial ¢;-embedding
step, and the only hurdle to the final proof of Theorem is the final low-stretch spanning
step. The latter we can handle with Remark [7.4.4] since minimum spanning tree can be
computed in parallel with Boruvka’s algorithm. We state the following corollary below to
be used in our parallel algorithms.

7.5 Vertex Sparsification and Recursion

This section is dedicated to proving the vertex-sparsification lemma, Lemma [7.3.15] restated
below:
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Lemma: Restatement of Lemma 7.3.15

Let G = (V, E) be a graph with n vertices and m edges, and let A be an algorithm
that inputs any vertex set S C V and outputs a (1 + 1/logn)-approximate S-SSSP
potential of G. Then, there is an algorithm that computes an ¢;-embedding of GG into
O(log®n) dimensions with distortion O(log®n) and calls A at most O(log?n) times,
plus O(m) additional work and polylog(n) additional time.

7.5.1 Case S ={s} of Lemma [7.3.15

In this section, we first prove Lemma [7.3.15| for the case when S = {s} for a single source
s € V in the lemma below. We then extend our result to any set S C V in Section [7.5.4]

Lemma 7.5.2: s-SSSP algorithm

Let G = (V, E) be a connected graph with n vertices and (n — 1) + ¢ edges, and let
a > 0 be a parameter. Let A be an algorithm that inputs a connected graph on at most
5t vertices and edges and outputs an a-approximate s-SSSP potential of that graph.
Then, for any source s € V', we can compute an a-approximate s-SSSP potential of G
through a single call to A, plus O(m) additional work and polylog(n) additional time.

Our approach is reminiscent of the j-tree construction of Madry [77], but modified to
handle SSSP instead of flow/cut problems[]

First, compute a spanning tree 1" of G, and let Sy C V' be the endpoints of the ¢ edges in
G — T together with the vertex s, so that |Sy| < 2t + 1. Next, let Tj be the (tree) subgraph
in T" whose edges consist of the union of all paths in T" between some pair of vertices in Sj.
The set Tj can be computed in parallel as follows:

1. Root the tree T arbitrarily, and for each vertex v € V, compute the number N(v) of

vertices in Sy in the subtree rooted at v.

2. Compute the vertex v with maximum depth satisfying N(v) = |Sp|; this is the lowest

common ancestor Ica(Sy) of the vertices in Sp.

3. The vertices in Tj are precisely the vertices v in the subtree rooted at Ica(Sy) which

satisfy N (v) # 0.

Let S3 be the set of vertices in Ty whose degree in Tj is at least 3, and let .S := Sy U S;.
Starting from Tj, contract every maximal path of degree-2 vertices disjoint from S into a
single edge whose weight is the sum of weights of edges on that path; let T} be the resulting
tree. Since every leaf in Tj is a vertex in Sy, and since every degree-2 vertex disjoint from S
is contracted, the vertex set of T} is exactly S. We furthermore claim the following:

SEssentially, according to the terminology of [77], any graph with (n — 1) + ¢ edges is a 5t-tree
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T7 has at most 4t vertices and edges.

Proof. Let ny, ng, and n>3 be the number of vertices in 77 of degree 1, 2, and at least 3,
respectively. Since every leaf in T} is a vertex in Sy, we have n; > |Sy|. Also, since T} is a
tree, it has ny + ny + n>3 — 1 edges, and since the sum of degrees is twice the number of
edges, we have

n1+2n2+3n23§2(n1+n2+n23—1) - n23§n1—2§ ‘SO|_2

The number of vertices in 7} is exactly n; + n>3, which is at most 2|Sy| — 2 < 4¢. The edge
bound also follows since T} is a tree. O

Let G be T; together with each edge in G —T added to its same endpoints (recall that no
endpoint in G — T is contracted). Since 77 has at most 4¢ vertices and edges by Claim m,
and since we add t additional edges to form G4, the graph G; has at most 4t vertices and 5t
edges.

Finally, let Gy be T together with each edge in G — T added to its same endpoints, so
that Gy is exactly G; with the contracted edges expanded into their original paths. Since
every edge in G — T is contained in G, we have that G — Gy is a forest. We summarize our
graph construction below, which will be useful in Section [7.5.4]

Lemma 7.5.4

Let G = (V, E) be a graph with n vertices and (n — 1) 4+ ¢ edges, and let T' be an
arbitrary spanning tree of G. We can select a vertex set V5 C V' and define the graph
Gy := G[Vy] such that (i) G — G is a forest, and (ii) we can contract degree-2 paths
from Gy into single edges so that the resulting graph G; has at most 4t vertices and
5t edges. The contracted edges in GGy have weight equal to the total weight of the

contracted path. This process takes O(m) work and polylog(n) time.

It is easy to see that the aspect ratio of Gy is O(M). Now, call A on G; with s as the
source (recall that s € Sy C S = V(Gy), so it is a vertex in Gy ), obtaining an SSSP potential
¢1 for G1. It remains to extend ¢; to the entire vertex set V.

7.5.2 Extending to Contracted Paths

First, we extend ¢; to the vertices (of degree 2) contracted from T to T;. More precisely,
we will compute a SSSP potential ¢y(v) on the vertices in Gy that agrees with ¢, on V(Gy).

Define ¢g(v) := ¢1(v) for v € V(G1), and for each such path vy, vq,...,v, with vy, v, €
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V(G1) we extend ¢g to vy, ..., v, as follows:

j -1
¢o(v;) := min {¢1(U0) + Z’w(vi—b v;), ¢1(ve) + Z w(v;, Ui+1)};

i=1 i=j

note that these values are the same if we had replaced the path by its reverse (vg, vo_1, ..., v0)
instead.

For all u,v € V(G;), we have dg,(u,v) = dg, (u,v).

Proof. Observe that any simple path P in Gy between u,v € V(G;) must travel entirely
along any path of degree-2 vertices sharing an edge with P. Therefore, for every contracted
path in G that shares an edge with P, we can imagine contracting that path inside P as
well. Since paths of degree-2 are contracted to an edge whose weight is the sum of weights of
edges along that path, the total weight of P does not change. Since P is now a path in G,
this shows that dg, (u,v) < dg,(u,v). Conversely, any path in Gy can be “un-contracted”
into a path in Gy of the same length, so we have dg,(u,v) < dg, (u,v) as well, and equality
holds. O

The vector ¢ is an a-approximate s-SSSP potential of Gj.

Proof. We first prove property (2). Since ¢g(v) = ¢1(v) for v € V(Gy), property (2) holds
for ¢y for edges GG1 that were not contracted from a path in Go. For an edge (u,v) that was
contracted, there is a contracted path vy, v1,...,v, where u = v; and v = v,y for some j.
First, suppose that

J J £—1
Po(vj) = ¢1(vo) + Zw(vifla v;) <= ¢1(vo) + Zw(viflyvi) < ¢1(ve) + Zw(vi, Vig1).
i—1 i—1 i—j

Then,

Go(vj41) — do(v) < (¢1(Uo) + jiiw(%—hvz)) - (¢1(Uo) + zj:w(vi—lavi)) = w(vj, vjt1)-

i=1 =1

Otherwise, if

-1 j -1
do(vj) = d1(ve) + Zw<vi7 vip1) = ¢1(vo) + Zw(vi—b v;) 2> ¢1(ve) + Zw<vi7 Vig1),
i—j i—1 i—j
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then

-1 =1
$o(vj+1) < dr(ve) + Z w (v, vit1) = d1(ve) + Zw<viavi+1) —w(vj,vj41) < 0.
1=j+1 1=j

Therefore, in both cases, ¢o(v) — ¢o(u) = ¢o(vj+1) — ¢o(v;) < w(vj,vj41). For the other
direction ¢g(v) — ¢o(u) < w(vj,vj41), we can simply swap u and v.

We now focus on property (1). Since ¢g(v) = ¢1(v) for v € V(G;), and since dg,(u,v) =
deg, (u,v) for u,v € V(Gy) by Claim [7.5.5, property (1) holds for u,v € V(Gy). We now
prove property (1) for vertices v ¢ V(Gy).

If v ¢ V(Gy), then v = v; for some path vy, vy, ..., v, contracted in Gy (vo, v, € V(Gy)).
Observe that dy := dg,(s,v0) + S1_, w(v;_1,v;) is the shortest length of any (simple) path
from s to v that passes through vy, and similarly, d, := dg,(s,v¢) + Zf:jﬂ w(vi_1,v;) 18
the shortest length of any (simple) path from s to v that passes through v,. Furthermore,
de,(s,v) = min{dy, d,}. We have

(61(0m) + Y wloios,9) = 01(6) = Y wlveess ) + - ey 5,0

1

Zw Vi—1, V4 +dG0<8 UO) = E?

=1

-.
Il

vV
AR

and similarly,

/-1 -1

1
(¢1(W) + Zw(’Uz‘, Ui+1)) —¢(s) > Zw Vi Vis1) + — - dg, (s, vr)
i=j i=j
-1
1 dy
> E(Zw Vi, Vin1) + da, (S, 'Ug)) =—
i=j

It follows that

%@%%U—m{@%+zwmwz@w+zw%mq

i=j
dy d
> mm{ 0, —K}
a’ «
1
——.d
o GO(SJU)J
proving property (1). ]
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7.5.3 Extending to Forest Components

It remains to extend ¢y to an SSSP potential in the original graph G. First, recall that all
edges in G — T have endpoints inside S = V(G1) C V(Gy), which means that G — E(Gy) is a
forest contained in T'. Moreover, since GoNT = Tj is connected, every connected component
(tree) in G — E(Gy) shares exactly one endpoint with V' (Gy) (otherwise, there would be a
cycle in T'). Therefore, any simple path between two vertices in V(Gj) must be contained in
Gyp. Since G is itself an induced subgraph of GG, in particular with the same edge weights,
we have dg(u,v) = dg,(u,v) for all u,v € V(Gy).

In addition, for each component (tree) C' in the forest G — E(Gj) that shares vertex r
with V(Gy) (which could possibly be s), any path from s to a vertex in C' must pass through
r. In particular, the shortest path from s to a vertex v € C' consists of the shortest path
from s to r (possibly the empty path, if » = s) concatenated with the (unique) path in C
from r to v. It follows that dg(s,v) = dg(s,r) + de(r,v).

With these properties of G' in mind, let us extend ¢ to the potential ¢ on V' as follows:
for v € V(Gy), define ¢(v) := ¢o(v), and for each connected component C' of G — E(G)
sharing vertex r with V(Gy), define ¢(v) = ¢o(r) + de(r,v). Since C is a tree, the values
de(r,v) for each v € V(C) are easily computed in parallel.

The vector ¢ is an a-approximate s-SSSP potential of G.

Proof. Since ¢y and ¢ agree on V(Gy), and since G[V(Gy)] and Gy agree on their edges
(including their weights), property (1) of Definition holds for all v € V(Gy) and prop-
erty (2) holds for all u,v € V(Gy).

Now fix a connected component C' of G — E(Gy) sharing vertex r with V(Gy). For each
vertex v € C, we have

(da(s,r) + de(r,v))
(s,0)

)
«

QI'—‘

$(v) = d(s) = (¢o(r) + dc(r,v)) = do(s) = — - dg(s,r) + de(r,v) >

> 1
!
d

proving property (1) for vertices in C. For property (2), consider an edge (u,v) in C. Since
C'is a tree, either do(r,u) = de(r,v) + w(u,v) or do(r,v) = do(r,u) + w(u,v), so in both
cases,

[6(u) — ¢(v)] = [(¢o(r) + de(r,u)) — (¢o(r) + de(r,v))| = [de(r,u) — de(r,v)| = w(u,v),

proving property (2). ]
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7.5.4 Generalizing to S-SSSP

Of course, Lemma requires calls to not just s-SSSP, but S-SSSP for a vertex subset
S C V. In this section, we generalize the algorithm to work for S-SSSP for any S C V.

Lemma 7.5.8: S-SSSP algorithm

Let G = (V, E) be a connected graph with n vertices and (n — 1) + ¢ edges, and let
«a > 0 be a parameter. Let A be an algorithm that inputs (i) a connected graph on
at most 70t vertices and edges with aspect ratio O(M) and (ii) a source vertex s,
and outputs an a-approximate s-SSSP potential of that graph. Then, for any subset
S C V, we can compute an a-approximate S-SSSP potential of G' through a single
call to A, plus O(m) additional work and polylog(n) additional time.

Let Gy and G be the graphs guaranteed from Lemma and let V), V7 C V be
their respective vertex sets. Consider the set C of connected components (trees) in G — Gy;
for each component C' € C, let r(C) be the vertex shared between C' and Vj, and let C
be the set of components C' with S N (V(C)\ {r(C)}) # 0. Root each component C' € C
at r(C), and let CT be the subgraph of C' induced by the vertices that have no ancestor
in SN (V(C)\ {r(C)}) (see Figure [7.5.4); we will first focus our attention on CT. Let
d(C) = do(r(C),SNV(C)) = det (r(C), SNV (CT)) be the distance from r(C) to the closest
vertex in S N V(C), which must also be in SN V(C).

Next, consider all paths P in G that were contracted into edges in C7; for each such
path P, let r1(P), ro(P) € Vi be the two endpoints of P. Let P be the paths P which satisfy
SN (V(P)\{ri(P),ra(P)}) # 0. For i = 1,2, let v;(P) be the vertex on P closest to r;(P),
and define d;(P) := dp(r;(P),v;(P)) = dp(r;(P),S N V(P)). Note that it is possible that
v1(P) = vyo(P), which happens precisely when |S N V(P)| = 1. Define PT to be the union
of the path from 71 (P) to v1(P) and the path from r5(P) to vy(P). Again, we first focus on
Pt

We construct a graph G, as follows. The vertex set is Vo := Vi U Upee V(CT) U
Upep V(PT) U {s} for a new vertex s. Add the graph G; onto the vertices V;, and for
each C € C and P € P, add the graphs CT and PT into V(C") and V(PT), respectively.
For each vertex v € SN Vj, add an edge of weight 0 between s and v, adding a total of
SN V| < |Vi| < 4t edges. Next, for each C' € C, add an edge from s to r(C') of weight d(C),
and for each P € P, add an edge from s to r;(P) of weight d;(P) for ¢ = 1,2. Since every
component C' € C has a distinct 7(C) € Vi, we have |C| < |V4| < 4t. Since every path P € P
gets contracted to a (distinct) edge in Gy, we have |P| < |E(G1)| < 5t. Therefore, we add
at most 13t edges from s.

Go has at most (|Va| — 1) + 14t edges, and for every vertex v € V5 \ {s}, we have
de,(s,v) > dg(S,v).
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Figure 7.3: Construction of the graph Gb.

Proof. Since G has (n — 1) + t edges, there exists some t edges ' C E such that G — F is
a tree. This means that G[V; \ {s}] has at most (|V2 \ {s}| — 1) edges in G — F, and since
|F| =t and we added at most 13t extra edges, G has at most (|V5 \ {s}| — 1) + 14¢ edges.

To prove the second statement, consider a vertex v € V5 \ {s}, and let P be the shortest
path from s to v in Gy. If the first edge on the path (adjacent to s) its other endpoint
(besides s) inside V; NS, then this edge has weight 0, and the path P minus that first edge
is a path in G; from S to v of equal weight. Then, for each edge on the path formed by
contracting a path in G to an edge in GG, we can expand the edge back to the contracted
path, obtaining a path the same weight in G.

Next, suppose that the first edge connects to vertex r(C') for some C' € C. In this case,
we replace that edge with the path in CT from SNV (CT) to r(CT) of weight d(C). The new
path is a path in G from V to v of the same weight, and we can expand contracted edges
as in the first case.

Otherwise, the first edge must connect to a vertex r;(P) for some P € P and i € {1,2}.
In this case, we similarly replace that edge with the path in PT from SNT(P?") to r;(PT) of
weight d(P), and the rest of the argument is analogous. ]

It is clear that G5 has aspect ratio O(M). We now apply Lemma on (G5 with source
s and algorithm A (note that 70t = 5-14¢), obtaining an s-SSSP potential ¢ on V5. W.l.o.g.,
we can assume that ¢9(s) = 0, since we can safely add any multiple of 1 to ¢o(s). We now
extend ¢ to V' by setting ¢o(v) := oo for all v € V' \ V5.

We next define a potential ¢¢ on V' as follows. For each C' € C, let ¢c(r(C)) := oo, let
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oc(v) = de(SN (V(C)\ {r(C)}),v) for v € V(C) \ {r(C)} (that is, ezxact distances in C
from S N (V(C)\ {r(C)}), and assume w.l.o.g. that ¢c(v) = 0 for all v € SN V(C) (see
Observation [7.2.10). For all remaining v € V' \ Ugee V(C), define ¢c(v) := oo. Similarly,
we define potential ¢p as follows. For each P € P, let ¢p(r1(P)) = ¢p(re(P)) := oo, let
op(v) :=dp(SN(V(P)\{r1(P),r2(P)}),v) for v € V(P), and assume w.l.o.g. that ¢p(v) =0
for all v € SN V(P); for all remaining v € V' \ Upp V(P), define ¢p(v) := co. Since each
C €C and P € P is a tree, this can be done efficiently in parallel as stated below, whose

proof we defer to Section [7.10.3]

Given a tree T = (V, E) and a set of sources S C V', we can compute an exact S-SSSP
potential in O(m) work and polylog(n) time.

Finally, define ¢(v) := min{¢s(v), ¢c(v), dp(v)}. Note that for all v € V, we have
¢i(v) >0 for all i € {2,C, P} by Observation [7.2.10} so ¢(v) > 0 as well.

The vector ¢ is an a-approximate S-SSSP potential of G.

Proof. Since ¢(s) = 0 for all s € S, property (0) of Definition holds. We now prove
property (1). Fix a vertex v € V, and suppose first that ¢(v) = ¢2(v) (i.e., the minimum
is achieved at ¢»(v)). Then, by Claim , we have dg,(s,v) > dg(s,v). This, along with
the guarantee ¢2(v) > dg,(s,v) from ¢, implies that ¢o(v) > Ldg,(s,v) > Ldg(s,v). Now
suppose that ¢(v) = ¢¢(v). Since ¢¢(v) = de(v, SNV (C)) for some C € C, we have

6(v) = de(v, SN V(C)) > de(v, SOV(C)) > de(v, S) > éd(;(v, s).

The remaining case ¢(v) = ¢p(v) is analogous, with every instance of C' and C replaced by
P and P, respectively.

We now focus on property (2). Note that if ¢o, ¢c, op each satisfied property (2), then by
Observation[7.2.11] ¢ would as well. In fact, a more fine-grained variant of Observation[7.2.1]]
states that for any edge (u,v) € E, if we have |¢;(u) —¢;(v)| < dg(u,v) for each i € {2,C, P},
then |p(u) — ¢(v)| < dg(u,v) as well. Hence, we only need to consider edges for which
|pi(u) — ¢i(v)| > dg(u,v) for some ¢ € {2,C, P}.

We first focus on ¢ = 2. Observe that by property (1) on ¢o, the only edges (u,v) € E
for which |po(u) — ¢o(v)| > dg(u,v) are those where ¢o(u) < oo and ¢2(v) = oo or vice
versall| Let us assume w.l.o.g. that ¢o(u) < 0o and ¢9(v) = oo; by construction, we must
either have u € SNV(CT) for some C' € C or u € SNV(PT) for some P € P. In the former
case, since ¢c(u) = 0 and ¢(u) > 0, we must have ¢(u) = 0, and since ¢¢(v) < oo and

6Let us assume for simplicity that co — oo = 0. To be more formal, we should replace each oo with some
large number M that exceeds the weighted diameter of the graph, so that we have M — M = 0 instead.
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®2(v) = pp(v) = o0, we also have ¢(v) = ¢¢(v). We thus have, for some C € C,

|6(u) = ¢(v)] = |¢c(u) = de(v)] = [0 = de(v)| = de(v) = do(v, SNV(C)) < da(u,v),

so edge (u,v) satisfies property (2), as needed.

Next, consider the case i = C. By construction, the only edges (u,r) € E for which
|bc(u)—pe(r)| > dg(u,v) are those where ¢¢(u) < oo and ¢¢(r) = oo or vice versa. Assuming
again that ¢¢(u) < oo and ¢¢(r) = 0o, we must have v € V(C') and r = r(C') for some C' € C.
By construction, we must have ¢(r) = ¢o(r) and ¢(u) = min{ps(u), ¢c(u)}. By property (2)
of @9, we have |po(u) — do(r)| < w(u,r), so it suffices to show that ¢c(u) > o(r) — w(u,r),
from which |p(u) — ¢(r)| < w(u,r) will follow.

By Observation and the fact that ¢(s) = 0, we have ¢a(r) < dg,(s,r). Also, by
construction of Gq, we have dg,(s,7) < wg,(s,7) = dao(S N (V(C)\ {r(C)}). Therefore,

ge(u) = de(S N (V(C)\{r(C)}),u) = dc(SN(V(C)\ {r(C)}),r) — w(u,T)
> da, (u, ) — w(u,r)
> ¢a(r) —w(u,r)

= ¢<T) - ’LU(U, T)?
as desired.
The case i = P is almost identical to the case i = C, except we now have r = r(P) or
r = r9(P). Since the rest of the argument is identical, we omit the proof. ]

7.6 Ultra-spanner Algorithm

In this section, we present our algorithm for constructing an ultra-spanner. It is a modifica-
tion of the weighted spanner algorithm of [82], where we sacrifice more factors in the spanner
approximation for the needed ultra-sparsity.

Lemma: Restatement of Lemma 7.3.14

Given a weighted graph G with polynomial aspect ratio and a parameter k£ > (1),
there is an algorithm to compute a k*-spanner of G with (n — 1) + O(™%52) edges in

O(m) work and polylog(n) time.

Our ultra-spanner algorithm closely resembles the weighted spanner algorithm of [82].
Their algorithm outputs an O(k)-spanner with O(n'*'/*logk) edges, which is not ultra-
sparse and therefore insufficient for our purposes. However, the log k factor of their algorithm
comes from splitting the graph into O(log k) separate ones, computing a spanner for each,
and taking the union of all spanners. We modify their algorithm to consider only one graph,
at the cost of an extra k-factor in the stretch, which is okay for our application. We first

introduce the subroutine for unweighted graphs from [82] (which dates back
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to [83]) and its guarantee, whose proof we sketch for completeness. Note that while this
algorithm can be adapted to the weighted setting, executing the algorithm efficiently in
parallel is difficult

Algorlthm 10 ESTCluster(G = (V, E), 8 € (0,1]), G is unweighted
: For each vertex u, sample Ou 1ndependently from the geometric distribution with mean
1/

2: Create clusters by defining C, := {v € V : v = argmin,cy d(v/,v) — d}, with ties
broken by a universal linear ordering of V. If v € C,, then w is the center of cluster C,
3: Return the clusters (', along with a spanning tree on each cluster rooted at its center.

For each edge in F, the probability that its endpoints belong to different clusters is at
most 3.

Proof. Fix an edge (v,v') € E, and let uy,us € V' be the vertices achieving the smallest and
second-smallest values of d(u', v) —§,, over all v’ € V| with ties broken by the linear ordering
of V. (In particular, v € C,,.) Let us condition on the choices of wu;, us and the value of
d(ug,v) — d0,,. First, suppose that u; < us in the linear ordering (that is, u; is preferred in
the event of a tie). Then, we know that

v e Cy, = d(up,v) — 0y, <d(ug,v') — 0, <= 04y > d(ug,v') — d(ug, V") + y,.

So far, we are conditioning on the event ¢, > d(uy,v) — d(ug,v) + d,,. By the memoryless
property of geometric variables, with probability 1 — 8, we have &, > (d(uy,v) — d(ug,v) +
du,) + 1. In that case, we also have

d(uy,v") =0y, < (d(ug,v)+1)—=by, < (d(ur,v)+1)—(d(uy,v)—d(ug, v)+0u,+1) = d(ug, v)+0u,,

so v’ € O, as well and edge (v, ") lies completely inside C,, .
If u; > wusy in the linear ordering instead, then we know that

veC, <= duy,v)— 0y, <dugv) =0, <= &y, > d(u,v)—d(ug,v) + Oy,

and the proof proceeds similarly.
Overall, for each edge in E, the probability that its endpoints belong to different clusters
is at most [. n

We now proceed to our ultra-spanner algorithm. Without loss of generality, the edge
weights of G range from 1 to W for some W = poly(n). For positive real numbers z and k,
define ||z ]|k := max{k®: o € Z, k* < z} as the largest integer power of k less than or equal
to x. Let ||G|lx = (V.| £]lx) be the graph G with the weight w(u, v) of each edge (u,v) € E
replaced by |w(u,v)||x, so that in particular, all edge weights in G' are now nonnegative
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integer powers of k. For each a € {0,1,2,...,[|[W]x}, define E, C || E||x as the set of edges
in |G|, with weight k*.

Algorithm 11 [Ultraspanner( |G| = (V, | E]x))
1: Initialize Hy () > H; C || E||x will be edges contracted over the iterations
2: Initialize ||.S||x < 0 > S|k C ||E]Jx will be the edges in the spanner
3: fora=0,1,2,...,[|[W], do
4
)

Let V=1, V1 ... be the connected components of H,
Let I', be the graph formed by starting with (V, E,) and contracting each V;*~* into
a single vertex
6: Run on the (unweighted version of) I',, with § := Cln” on I, for suffi-
ciently large C, and let F' C I', be the forest returned
7 Update ||S||x < S]] U F
8: Set Hy «+— Hy,_ 1 UF
9: Add to || S|k all edges e in I', whose endpoints lie in different connected components
of F
10: return ||.S||; as the spanner

Lemma 7.6.3

If Ultraspanner| succeeds (for a notion of success to be mentioned), then the output
S]] is a k-spanner with at most n — 1 + O(™%8") edges. We can define our success
condition so that it happens with probability at least 1/3, and that we can detect if
the algorithm fails (so that we can start over until it succeeds). Altogether, we can run
the algorithm (repeatedly if necessary) so that w.h.p., the output ||S||x is a k-spanner
of |G|y with at most n— 1+ O(mlog") edges, and it takes O(m) work and O(k) time.

Proof. We say that [Ultraspanner| fails if on any iteration «, some 4, in the computation of
ESTCluster|satisfies §, > k/6. Observe that if the algorithm does not fail, then every call to
ESTCluster| takes O(k) time (and O(m) work) and returns clusters with diameter at most
2-k/6 = k/3. We now bound the probability of failure: for any given vertex v in some I',,
the probability that §, > k/6 is e ##/0 = ¢=(C/6)Inn — p=C/6 There are at most n vertices
in each I',, and at most [log, W | + 1 = O(1) many iterations since G has bounded aspect
ratio, so taking a union bound, the failure probability is at most O(n‘C/ 61,

Assume now that [Ultraspanner| does not fail. Then, we prove by induction on « that
the diameter of each connected component of H, is at most k**!. This is trivial for o = 0,
and for a > 0, suppose by induction that the statement is true for a — 1. Since the algorithm
does not fail, returns clusters with (unweighted) diameter at most k/3. In the
weighted T, these clusters have diameter at most k/3 - k* = k®*1/3. Observe that H, is
formed by starting with these clusters and “uncontracting” each vertex into a component
of H,_1. By induction, each component in H,_; has diameter at most k“. Therefore,
between any two vertices in a common component of H,, there is a path between them
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consisting of at most k/3 edges of length k* and at most k/3 + 1 subpaths each of length
at most k, each inside a component in H, ;. Altogether, the total distance is at most
k/3-k*+ (k/34 1)k~ < k! (assuming k > 3).

Let us now argue that the stretch of ||S||x is at most k if the algorithm does not fail.
Observe that the edges added to || S| in line |§|on an iteration « are precisely the edges whose
endpoints belong to different clusters in the corresponding call. Conversely, any
edge e € E, not added to ||.S||x have both endpoints in the same cluster. By the previous
argument, this cluster has diameter at most k™! assuming that the algorithm does not fail.
Therefore, the stretch of edge e is at most k.

Finally, we bound the number of edges in the output S. By Lemma [7.6.2] for the
call on iteration «, every edge in E,, has its endpoints in different clusters with
probability at most 3, which is when it is added to ||.S||x in line @ Over all iterations «, the
expected number of edges added to || S| in line @ is at most fm. By Markov’s inequality,
with probability at least 1/2, there are at most 2m edges added in line @ If this is not the
case, we also declare our algorithm to fail. Note that the failure probability now becomes
at most 1/2 4+ O(n=¢/%+1) < 2/3 (for C large enough). Moreover, it is easy to see that the
edges added to S on line [7] form a forest, so at most n — 1 are added there. Altogether, if
the algorithm succeeds, then there are at most (n — 1) +26m = (n — 1) + O(%) edges
in the output, which is a k-spanner.

Lastly, [U1traspanner| can clearly be implemented to run in O(m) work and O(k) time.
Moreover, we only need to repeat it O(logn) times before it succeeds w.h.p. O

Using Lemma [7.6.3] we now prove Lemma [7.3.14] as follows. Let S C E be the cor-
responding spanner in G sharing the same edges as || S|y C || £|lx (with possibly different
weights). Intuitively, since ||G]|; approximates the edge weights of G up to factor k, the
k-spanner S should be a k*-spanner on G. More formally, given an edge (u,v) € F, let
u = vy, v1,...,v = v be the shortest path in || S]|y. We have

¢ ‘
ds(u,0) <Y dg(vimy,v) <Y k- dysy, (vim1,0;) = k- dysy, (u,v) < k- kdjey, (u,v)
i1 i—1

< k2dg(u,v).

Therefore, S is a k?-spanner of G.

7.7 Sherman’s Framework via Multiplicative Weights

In this section, we provide a self-contained proof of Theorem [7.4.2] using the multiplicative
weights updates framework at the loss of an additional log(n/¢€), which can be disregarded
in our parallel algorithms.
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Theorem 7.7.1: Weaker version of Theorem 7.4.2

Given a transshipment problem, suppose we have already computed a matrix R satis-

fying:
1. For all demand vectors b € R",

opt(b) < 1Rb < £ - opt(b) (7.10)

2. Matrix-vector products with R and R? can be computed in M work and

polylog(n) timd]
Then, for any transshipment instance with demand vector b, we can compute a flow
vector f and a vector of potentials ¢ in O(k?(m + n + M) e?) time that satisfies:

L ||Cf]l, < (1 + €T < (1+ €) opt(b)
2. opt(Af —b) < Bopt(d)

®M can potentially be much lower than the number of nonzero entries in the matrix R if it can
be efficiently compressed.

J

We begin with the following classical result on solving linear programs approximately

Theorem 7.7.2: Solving LPs with Multiplicative Weights Update

with multiplicative weights update framework. We state it without proof, since the result is
a staple in advanced algorithms classes.

Let 6 < 1 and w > 0 be parameters. Consider a convex set K C R", a matrix
M € R™™ and a vector ¢ € R™. (We want to investigate approximate feasibility of
the set {y € K : My < c}.) Let O be an oracle that, given any vector p € A,,, either
outputs a vector y € K satisfying p" My < p’c and ||[My — ||, < w, or determines
that the set {y € K : p’ My < pTc} is infeasible. Then, consider the following
algorithm:
1. Set Po < %]]. SAV.
2. Fort =1,2,...,T where T = O(w? logm/§?):
(a) Call oracle O with p,_; € A,,, obtaining vector y(*)
(b) If O determines that no y € K exists, then output p, ; and exit

(c) For each j € [m], set wj(t) — wj(t*l) cexp(d - (My® — ¢);) = exp(6 -
Zie[t](My(l) —c)j)
(d) For each j € [m], set pg-t) — wj(»t)/ > icim] w,ft)

3. Output %Ziem y® e K
If this algorithm outputs a vector p € A,, on step 2(b), then the set {x € K : Mz < ¢}
is infeasible. Otherwise, if the algorithm outputs a vector x € K on step 3, then we
have Mz < ¢+ 41.
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Lemma 7.7.3

Consider a transshipment instance with demands b and a parameter ¢t > opt(b)/2.
Let 7 be a parameter, and let R € RI*YV be a matrix satisfying (7.10) for some
parameter k. Then, there is an algorithm that performs O((x/€)? logn) matrix-vector
multiplications with A, AT, R, and RT, as well as O((x/€)? log n) operations on vectors
in R", and outputs either

1. an acyclic flow f satisfying 1C'f <t and 1RAf — Rb < €t, or

2. a potential ¢ with value b7 ¢ = t.

We will run Multiplicative Weights Update (Theorem [7.7.2)) to determine feasibility of
the region

{yeR": HyTRAC’_lHOO + %yTRb < —eand |yl <1},

which is modeled off the dual LP formulation for transshipment; this connection will become

clearer once Claim is proved.

We set ¢ := €l and K := {y € R" : ||y||, < 1} in Theorem [7.7.2l As for matrix M, the
constraint ||yTRAC’*1 HOO + %yTRb < —e can be expanded into

1
+y"RAc; X, + EyTRb < —e Ve€eF,

so the rows of matrix M consist of the 2m vectors (£RAc;'x. + $Rb)” for each e € E.

We now specify the oracle O. On each iteration, the algorithm of Theorem[7.7.2] computes
values pf, p; > 0 for each e € E satisfying > _(pS + p, ) = 1. The oracle needs to compute
a vector y satisfying ||y||. < 1 and

1 1
> (pj (y"RAc X, + ;yTRb) +p7 (~y"RAC X + ;yTRb)) < —e
eckE
This inequality can be rewritten as
1
T 1+ -
RA e — e) + = < —e.
y ( > e v xe — poxe) + tRb) < —e
eclE
Observe that a solution y exists iff
1
1RA T xe — poxe) + —Rb > €, 7.11
eeZEce (Pexe = pexe) + 7 Rb =€ (7.11)

and if the inequality is true, then the vector y := —sign(RA Y, . ¢ ' (0F xe — P2 Xe) + %Rb)
is a solution, so the oracle outputs it.
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We set the error parameter § to be €/(2x). Then, either the algorithm of Theorem [7.7.2]
outputs p that violates ([7.11)) on some iteration, or after a number of iterations (depending
on p, which we have yet to bound), the average of all vectors y computed over the iterations
satisfies

1 1
£y RAC X+ 7y RbS —e+ 5= =2 Ve€ B = [[y"RACT| + 1y Rb< -3,
(7.12)

First, suppose that the second case holds:

Suppose Theorem outputs a vector y satisfying (7.12)). Then, we can compute a
potential ¢ satisfying condition (2).

Proof. Consider the vector ¢ := —(yT R)T. We have
loac |~ teth< - <o
0 o 0TS 2 T
In particular, $¢§b > 0. Let ¢ be the vector ¢ scaled up so that $¢”b = 1. Then,
T -1 L r T A1 L7
|o"AC ||Oo—¥¢> b<0 = |¢"AC Hoo<¥¢ b=1.

Since ¢ satisfies the transshipment dual constraints, it is a potential. Moreover, ¢*b = t, so
¢ satisfies condition (2). O

Let us now consider the first case:

Claim 7.7.5

Suppose Theorem outputs values p}, p, > 0 satisfying > _(pf +p.) =1 and

1
ILRAZ o PFxe — P Xe) + ;Rb < €.

eeE

Then, we can compute an acyclic flow satisfying condition (1).

Proof. Let us construct a flow f € R” defined as f := —tY__ . (pSxe — P2 Xe). We have

ICf =1—t> (pixe —poxe) ==t > _|p5 —po | <> (pF +p.) =t

ecE eckE eckE

and
1-RAf + Rb < €t;
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it remains to show that f is acyclic. Fix an edge e = (u, v), so that Ax. = xu — Xo- We have
that f flows from u to v iff

fe>0 <= —tc'(pd —p) >0 <= pf <p,.

By the construction of p¥ from Theorem [7.7.2]

1 ; 1 .
pi<pe = ) ((RA X+ SRy —e) < S ((—RAG 'y + SRy — )

i€lt] i€t]

— (RAc;lxe)TZy(i) < (—RAcglxe)TZy(i)

i€(t] i€[t]
Let 7:= >y y®, so that this becomes

(RAcglxe)Tg < (—RAce_lxe)Ty — @TRce_l(Xu — Xo) < —@TRce_l(Xu - Xo)
= yTchlxu < @TRce_lxv.

Therefore, f will only flow from u to v if (g* R), < (" R),; it follows that such a flow cannot
produce any cycles. O]

We now bound the value w needed for Theorem [7.7.2] which in turn bounds the number
of iterations 7.

In Theorem [7.7.2] we can set w := 3k.

Proof. Consider an iteration where Theorem outputs values pf,p-, and define f :=
—t >, o (pxe — P2 xe) as before, so that 1C'f < ¢. Let f* be an optimal flow for demand
vector b, and by assumption, 1C f* = opt(b) < 2¢t. We have
1RAf — Rb=1RAf" — Rb+ RA(f — f7)
= 1RA(f — f7).

The flow f — f* has cost 1C(f — f*) < 1Cf + 1Cf* < t + 2t = 3t and satisfies demand

LRAf — Rb = IRA(f — f*) < 5 opt(A(f — f*)) < -3,

which 1mphes that
1RA E ¢ 1(p Xe b Xe) ! b <— 3K.
e e e t‘R K

eckE
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Since any vector y the algorithm chooses must satisfy ||y|| ., < 1, we must have

1
YT (RAZ co (PE Xe — P2 Xe) + ;Rb) < 3k,

eckE

so w = 3k works, as promised. O

Remark 7.7.7

We only used the upper bound in ([7.10]); the lower bound will become useful when we
work with condition (1) in the lemma. Moreover, we will not need that the flow f is
acyclic, but this property may be useful in other applications.

We now claim that Theorem [7.7.1] follows from Lemma [7.7.3] We apply Lemma [7.7.3]
O(log(n/e€)) times by binary-searching on the value of ¢, which we ensure is always at least
opt(b)/2 as required by Lemma [7.7.3] Begin with ¢ = poly(n), an upper bound on opt(b).
First, while Lemma outputs a flow f (case (1)), set ¢ «— 1< for the next iteration. We
claim that the new ¢ := 1<t still satisfies ¢' > opt(b)/2:

opt(h) < 1f + opt(Af —b) < 1f + 1RAf — Rb < t + et —> %@ < (%)t:t’.
Repeat this until Lemma outputs a potential ¢ instead, which signifies that opt(b) >
bT¢ = t. At this point, we know that opt(b) € [t,2t], and we can properly run binary search
in this range, where a flow f returned for parameter ¢ signifies that opt(b) < ¢t + et, and a
potential ¢ returned means that opt(b) > ¢. Through binary search, we can compute two
values ty,t, such that ¢, —t, < eopt(b) and opt(b) € (ts,1,).

Then, run Lemma with parameter ¢t = t,/(1 + €). We claim that we must obtain a
potential ¢, not a flow f: if we obtain a flow f instead, then

opt(b) < 1f +opt(Af —b) < 1f+ 1RAf — Rb <t+et =t

contradicting the assumption that opt(h) > #,. This potential ¢ satisfies bT¢p = X

1+e —
tT_ii':t(b) > °pt(b);:€°pt(b) = (1 — O(e))opt(b), which is almost optimal. At this point, we can
compute a (1 + €)-approximation of opt(b), but we still need a transshipment flow f.

Next, run Lemma [7.7.3| with parameter ¢ = ¢,; we claim that we must obtain a flow f this
time: if we obtain a potential ¢ instead, then opt(b) > b7 ¢ = t,., contradicting the assumption
that opt(b) < ¢,. This flow satisfies 1 f < ¢, < t,+eopt(b) < opt(b) +eopt(b) = (1+¢)opt(b).
However, we are not done yet, since f does not satisfy Af = b; rather, we only know
that opt(Af —b) < 1R(Af —b) < eopt(b). The key idea is to solve transshipment again
with demands b, := Af — b; if we can obtain a (1 + €)-approximate flow f; satisfying
1f; < opt(by) = opt(Af —b) < eopt(b) and TR(Af; —by) < eopt(h;) < e?opt(b), then the
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composed flow f + f; has cost

If+fi<1f+1f <(1+4¢€)opt(h)+ (1+e€)opt(by) < (1+ €)opt(b)+ (1 + €)eopt(d),

which is (1 + O(e))opt(b). We can continue this process, defining b; := Af;_; — b;_; and
computing a flow f; satisfying 1 f; < opt(b;) = opt(Af;_1 —b;_1) < eopt(b;_1) < €'opt(b) and
1R(Af; — b;) < eopt(b;) < e lopt(b) and adding it on to f + f1 + fo+ -+ fi_1. Assuming
e < 1/2, say, we can stop after T := O(logn) iterations, so that the leftover demands by,
satisfies opt(bri1) < %opt(b). At this point, we can simply run an n-approximate algorithm
to demands by ; by routing through a minimum spanning tree (see Remark, computing
a flow fry, satisfying 1fr.1 < n - J5opt(b) < eopt(b), assuming € > 1/n?. (If € = O(1/n?),
then a transshipment algorithm running in time O(1/€%) > O(n?) is trivial.) The final flow
f+ fi+ fo+---+ fry1 has cost at most (1 + O(e))opt(b).

We have thus computed ¢ satisfying 1f < (1 + O(e))opt(b) < (1 + O(€))b? ¢, so (f,®)
is an (1 + O(e))-approximate flow-potential pair. Finally, we can reset € a constant factor
smaller to obtain a (1 + €)-approximation. This concludes the algorithm of Theorem [7.7.1}
it remains to bound the running time.

In each iteration of Theorem [7.7.2] we perform O(1) matrix-vector multiplications with
A, AT R, and RT, as well as an additional O(m) work and polylog(m) time, and the same
holds for the oracle O. This requires O(n 4+ m + M) total work. By Theorem [7.7.2] there
are O(w?logm/8%) = O((r/€)*logn) iteration inside Lemma[7.7.3 to compute one flow f or
potential ¢. Finally, Lemma is called polylog(n) times as described above, hence the
promised running time.

7.8 Transshipment to Expected SSSP: Sequential

In this section, we devise an algorithm that solves the approximate expected single-source
shortest path problem, defined below, using multiple sequential calls to approximate trans-
shipment. The fact that the recursive calls are made sequentially does not immediately
imply a parallel algorithm, but in Section [7.8.1) we show how to save enough computation
between the recursive calls to ensure a parallel algorithm. This extra step is more technical
than insightful, hence its deferral to a separate subsection.

Finally, in Section[7.9) we show how to reduce SSSP to this expected version of SSSP [14].
Together, Sections and form a complete proof of Theorem We remark that
while Section is simply a rephrasing of a similar routine in [I4] and only included for
self-containment, this section is novel, albeit still inspired by [14].

161



Definition 7.8.1: Approximate expected s-SSSP Tree

Given a graph G = (V| E), a source s, and a demand vector b satisfying b, > 0 for
all v # s, an a-approximate expected s-SSSP tree is a randomized (not necessarily
spanning) tree T satisfying

Z b,U-dT(s,U)] <« Z by - dg(s,v).

v:by >0 0:by >0

E

If b, > 0 for all v # s, then the tree 7" must in fact be spanning. We also remark that
the term expected has two meanings here. First, the tree T is randomized, so the guarantee

Z by - dr(s,v) < « Z by - dg(s,v)

v:by >0 v:by >0

is only satisfied in expectation. However, even if this guarantee is satisfied with probability
1, the distances dr(s,v) are not automatically a-approximate distances for every v; rather,
they only hold on average (weighted by b,). Note that in the exact setting a = 1, all distances
dr(s,v) are indeed exact, but this property breaks down as soon as «a > 1.

Define Wggssp(n, m, «) as the work to compute an a-approximate expected SSSP with
arbitrary demand vector b satisfying b, > 0 for all v # s. Our algorithm [ESSSP] is itself
recursive and satisfies the following recursion:

Lemma 7.8.2

Wesssp(n, m, (1 + 3¢)a) < Wrs(m, €) + Wgsssp(n/2, m, «).

Of course, we can solve expected SSSP exactly (o = 1) in constant time on constant-sized
graphs, so this recursion has depth at most log, n. Unraveling this recursion, the algorithm
calls transshipment at most log, n times, and the error (1 + 3¢) blows up multiplicatively
over each recursion level, obtaining

Wesssp(n, m, (1 + 3¢)1°82™) < log, n - Wirg(m, €) + O(m).
Resetting the value €, we can rewrite it as
Wesssp (n,m, 14+ €) < O(logn) - Wig(m, ©(e/ logn)) + O(m), (7.13)

which is our targeted recursion for our algorithm below.

Throughout the section, fix a (1 + €)-approximate transshipment flow f satisfying the
given demand vector b (with b, > 0 for all v # s). The key insight in our analysis is to
focus on a random walk based on a slight modification of the transshipment flow f. Define
a digraph @ = (VU{L}, B,zﬁ) as follows: start from G by bidirecting each edge of E,
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Algorithm 12 ESSSP(G = (V, E), s,b, (1 + 3¢)a)

Assumption: demand vector b satisfies by > 0 and by < 0 for all t € V' \ s

1:

Compute a (1 + €)-approximate transshipment f on G with demand vector b

2: Initialize the digraph A + ()
3: Every vertex u € V \ s with in(u) # (0 independently samples a random neighbor

v € out(u) with probability f(u,v)/fou(u) and adds arc (u,v) to
Add a self-loop (s, s) of zero weight to

5. Let A be the undirected version of

10:

11:

12:
13:
14:
15:

16:
17:
18:

19:
20:
21:
22:

23:
24:

25:

Initialize G’ < (0, ) as an empty undirected graph > Graph to be recursed on, with
< n/2 vertices
Initialize b’ as an empty vector > Demands to be recursed on
for each connected component C of A do
c(C) « total weight of edges in the (unique) cycle in C' (possibly the self-loop (s, s))
Let T be the graph C' with its (unique) cycle contracted into a single vertex ro >
Te is a tree
Add a vertex vc to G, and set demand b, <= > (o) bo

for each edge (u,u’) in E do

Let C and C' be the connected components of A containing u and u’, respectively

if C # (' then

Add an edge between ve and ver with weight w(u, u') +dr, (u, 7¢) +dg,, (W', ror) +

c(C) 4+ ¢(C")
Let s' < ve,, where Cy is the component of A containing s
Collapse parallel edges of G’ by only keeping the parallel edge with the smallest weight
Recursively call [ESSSP[G’, s, 1/, @), obtaining an a-approximate expected SSSP tree 1"
of G’
Initialize T' <+ () > The expected SSSP tree
for each edge (v,v’) in 7" do

Let (u,u’) € E be the edge responsible for adding edge (v,v’) to G’

Add edge (u,u’) to T
for each connected component C' of A do

Remove an arbitrary edge from the (unique) cycle inside C', and add the resulting
tree to T'
return 7'
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keeping the same weight in both directions. Then, add a new vertex 1 and a single arc
(s, L) of weight 0. Let C' denote the diagonal matrix indexed by ﬁ, where diagonal entry
(u,0),(u,0) 15 the cost of arc (u,v) € E under the weights .

Next, define a flow f on digraph 8 as follows: for each edge (u,v) € E, if fy,) > 0, then
add f(y,) flow to f along the arc (u,v), and if fr, . < 0, then add — f(,,,) flow to f along
the arc (v,u). Lastly, add —b, flow to f along arc (s, L). Observe that this flow satisfies the
demands b, for each v € V'\ s, demand 0 for s, and demand b, for L. Moreover, the cost
18]‘ of the flow f equals 1C'f.

—

For each vertex v € V, define in(v) := {u e : f(u,v) > 0} as the neighbors of v
that send flow to v, and define out(v) := {u € V : f(v,u) > 0} as the neighbors of v
that receive flow from v. For convenience, define fi ( ) = D uein(y /(W v) and fou(v) =

Zueout(v) f(UJ U’)

Define b*(v) := max{b,, 0}, so that b*(s) = 0 and b*(v) = b(v) for all v # s. Define
V* C V as the vertices t for which there exists a ¢ — L path using only arcs supported by
the flow f (that is, arcs (u,v) with f(u, v) > 0). We will only be considering vertices in V*
for the rest of this section.

For all vertices t € V', if b(t) = b (¢t) > 0, then ¢ € V*. Moreover, for all v € V*U{ L},
we have in(v) € V* U {L} and out(v) € V*U{L}. In other words, the vertices in
V*U{L} are separated from the vertices in V' \ V* by arcs supported by f.

Proof. For the first claim, let R C V U {L} be all vertices reachable from ¢ along arcs
supported by f ; we need to show that L € R. Since there are no arcs in f going out of R,
by conservation of flow, we must have > _.b(v) < 0. Since t € R and b*(t) = b(t) > 0,
we have 3 p\,b(v) < 0. But the only vertex in V' U {L} with negative demand is L, so it
must hold that 1 € R, as desired.

We now prove the second statement. If suffices to show that there are no arcs between
V*U{L} and V \ V*. There cannot be an arc (u,v) supported by f with u ¢ V* U {1}
and v € V*U{L}, since that would mean u can reach L by first traveling to v. Suppose for
contradiction that there is an arc from V* U {L} to V' \ V*. Since there is no arc the other
way, by conservation of flow, it must follow that >~ oy, b(v) < 0. But the only vertex L
with negative demand is L, so it cannot be that > .y ¢, b(v) <0, a contradiction. O

For each vertex t € V*, consider the natural random walk from ¢ to L in 87 weighted by
the flow f start from ¢, and if the walk is currently at vertex u € V, then travel to vertex
v € out(v) with probability f(u, v)/fou(u) (independent of all previous steps); stop when L
is reached. Let this random Walk be the random variable W;, which is guaranteed to stay
within V*U {L} by Claim

Given a walk W in 8 deﬁne length(1W) to be the length of the walk under the weights
w. In particular, length(W};) is the length of the random walk W; from ¢ to L.
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The claim below relates the transshipment cost to the expected lengths of the random
walks W, for t € V*. This allows us to later charge our expected SSSP distances to the lengths
of these concrete random walks, which are easier to work with than the transshipment flow
itself.

Claim 7.8.4
We have

S b (¢) - Ellength(W)] < 1C f.

eV
In other words, if, for each t € V*, we sample a random walk from ¢ to L. and multiply
its length by b*(¢), then the sum of the multiplied lengths over all ¢ is at most ]18 f
in expectation.

. J

Proof. For each vertex v € V*U{.L}, let freq,(v) be the expected number of times v appears
in W;. We first prove the following:

Proof. For each t € V*, treat E[freq,(v)] as a function from V*U{L} to R>, which satisfies
the following equations:

Effreq,(t)] = 1+ > Elfreq,(u)] - = = (7.14)
u€in(t) out (U
Effreq,(v)] = Y Elfreq,(u)] f“’(“; Yoe (V U{Lh\t (7.15)
u€in(v) out (U

Note that the equations are well-defined, since by Claim , all vertices u € in(t) are in
V*ift € V*. Define the function f(v) := >, . b*(v) - E[freq,(v)], so our goal is to show
that f(v) = fou(v) for each v 6 V*. We sum Equations and as follows: for
each ¢, multiply Equations (7.14)) and - ) by b7 (t), and then sum over all ¢, obtaining

f(v) + > fu)- = (v Yo e V*U{L} (7.16)
u€in(t) fut(u)

L)y=> bt (7.17)

teV*

K‘31

We first show that the solution f(v) = fou(v) for v € V* and f(L) = > ey T (t) satisfies
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the above system of equations. Equation (7.17) is clearly satisfied, and for ([7.16]), we have

—

Jout(0) = 55 @0) + fu) =67 @) + 3 Fluv) =t @)+ Y ﬁ“t(“)'f(ulvi‘
u€in(v) u€in(v) out (U

We now claim that there is a unique solution to f, which is enough to prove the claim.
Suppose there are two solutions f and f’ that satisfy (7.16)) and (7.17). Let g be their
difference: g(v) := f(v) — f'(v) for all v € V*, which satisfies

gv) = Z g(u) - _.(uét)) Vo e V*U{Ll} (7.18)
u€in(t) out (U
g(L) =0 (7.19)

We want to show that g(v) = 0 for all v € V*. It suffices to show that g(v) < 0 for all
v € V* since Equations ([7.18)) and ([7.19)) are satisfied with g(v) replaced by —g(v), so we
would prove both ¢g(v) <0 and —g(v) < 0, which would give g(v) = 0.

To show that g(v) < 0 for all v € V*, let v* := argmax,ey+ g(v). By (7.18), g(v*) is a
weighted average of g(u) over vertices u € in(v*), so

o flu, o). 2
g(v") ug(;*)g(u ﬁmt 2(: o =

so the inequality must be satisfied with equality, and g(u) = g(v*) for all u € in(v*).
Continuing this argument, any vertex u for which there exists a (possibly empty) v — v*
path in f satisfies g(u) = g(v*). Define R := {u € V* : exists u — v* path in f} as these
vertices. Suppose for contradiction that g(v*) > 0, or equivalently, g(u) > 0 for all u € R.
Then, summing over all v € R, we obtain

20 =2 2

vER vER u€in(v

f v)
_Z Z out()

vER u€in(v)NR

€R R:u€in(v)
g(u)>0 g(u) o
< 7.20
5 (3 o) (720
ueR veV*



Since v* € V*, there exists a v* — L path in f Since v* € R and L ¢ R, there exists an
arc (u/,v') on the path with v/ € R and v/ ¢ R (and f(u/,v') > 0). Consider the inequality
at - The inequality holds for each u € R in the outer summation, but for u = u’ in
particular, we have 37 p. i ) < Y veve flu,v). Since g(u) > 0 by assumption, the

inequality at ([7.20]) is actually strict, which gives the contradiction ) . g(v) < >, crg(u).
It follows that g(v) < g(v*) <0 for all v € V*. o

We now resume the proof of Claim [7.8.4] For all ¢t € V*, by linearity of expectation,

E[length(17;)] Z freq,(u Z f u, ) w(u,v).

ueV* vEout(u) fout U)

For each ¢ € V*, multiply the equation by b"(¢), and sum the equations, obtaining

Z bt (t) - E[length(W;)] Z bt (t Z freq, (u Z f{uv) B(u,v)

teV* teVv* ueV* vEout(u) OUt<
_ + f
= Z b™(t) - freq,(u Z wW(u,v)
ueV* teVv veout(u out

vEout(u) OUt( )

This concludes Claim [7.8.4] O

We remark that all our claims so far are in expectation, and therefore do not care about
dependencies between the walks W, for different t € V*.

For each walk W;, we can imagine sampling it as follows: for each vertex u € V*, sample
an infinite sequence of arcs (u,v) for v € out(u), each independent of the others and with
probability f(u, v)/ fou(u); let these arcs be ¥, e, . ... Once the arcs are sampled for each
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u € V*, the random walk W, is determined as follows: start at ¢, and if the walk is currently
at u € V*, then travel along the next unused arc in the sequence e}, e, ...; in other words,
if we have visited vertex u on the walk k times before the current visit, then travel along the
arc ey ;. It is easy to see that the distribution of this random walk is exactly W;.

Let us first sample the set of sequences e}, e, ... for each u € V*, and then determine the
walks W, using this set of sequences for all ¢; note that the walks W, are heavily dependent
on each other this way. Furthermore, observe that the arcs {e} : u € V*} are distributed the

same way as the arcs in X from [ESSSP,
Lemma 7.8.6

Suppose we execute line 3| of so that each vertex u has (independently) sampled
a neighbor v* and added arc (u,v") is added to A. Let E be the event that in the
infinite sequences ey, ey, ..., we have e¥ = (u,v") for all u € V*. Let opt = opt,(b) be
the optimum transshipment cost with demands b in G, and let opt’ = opt(b') be the
optimum transshipment cost in the recursive call at line Then, by conditioning on
E in the random walks W;, we obtain

> b (t) - Ellength(W;) | E] + 2e-opt > B | > (b*(¢) - dr,, (u, rc))'E + opt’.

teV* teV*

(7.21)

Proof. Consider the following “greedy” cycle-finding algorithm for walks: given a walk W,
travel along the walk in the forward direction, and whenever a cycle is found, immediately
remove the cycle; output the set of all cycles removed. Let cycles(WW;) be the total length of
all cycles removed, where length is measured by the weights . We first show that cycles(WW})
must be small compared to opt, so that we can later charge to arcs in these cycles.

Subclaim 7.8.7

Given a walk W, let cycles(W) be the total length of cycles computed by the
cycle-finding algorithm, where length is measured by the weights @. Recall that
the transshipment flow f is a (1 + €)-approximation of the optimum opt (that is,
]lﬁfﬁ (1 + €)opt); then, we have

Z bt (t) - Elcycles(W;)] < € - opt.

teV*

Proof. For each walk W, sampled, remove the cycles computed by the algorithm to obtain
another walk W}, still from ¢ to L. Then, let W, be the walk W/ minus the last vertex L,
whose new last vertex must be s. The flow obtained by sending b*(¢) flow along the walk
W, for each t € V*\ s is a transshipment flow satisfying the demands b*(¢) for each t # s.
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Therefore,

opt < Z bt (t) - E[length(W, )]
teV*\s
= Z bt (t) - E[length(W})]
teV*\s
< ) b(t) - Ellength(1W))]
tevs
= Z bt (t) - E[length(W;) — cycles(W;)]
tev=
< Z bt (t) - E[length(W;)] Z bt (t) - Elcycles(W;)]
tev tevs
Clm.[F.84
< 18f Z bt (t) - Elcycles(W;)]
tevs
< (1+e€opt — ¥ b (t) - Elcycles(W;)],
tev
and rearranging proves the claim. o

Let us return to proving (7.21]). We actually show that for any set of infinite sequences

{et ey, ... u € V*} satisfying event E (that is, et = (u,v") for all u € V*), we have
Z bt (t) - (length(W;) + 2cycles(W;)) Z b*(t) - dr.(u,rc) + opt’. (7.22)
tev= tev*

Assuming ([7.22)), taking expectations and then applying Subclaim m gives

Zb+ E[length(W;) | E] 4 2¢ - opt
tev
> Z b*(t) - Ellength(W;) | E] + 2 Z bt (t) - Elcycles(W;) | E]
tev tev
= > b*(t) - (Ellength(W,) | E] + 2E[cycles(W,) | E)
tev*
> E[b"(t) - dr.(u,rc) | E] + opt/,
as desired.
For the remainder of the proof, we will show (7.22)) given any set of arbitrary infinite
sequences {el ey, ... : u € V*} satisfying e} = (u,v*) for all u € V*. In particular, now

that we have fixed these infinite sequences, all randomness goes away, so there are no more
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probabilistic arguments for the remainder of the proof.

For each t, we can view the random walk W, as follows: for each vertex u € V*, the first
time the walk reaches w, it must travel along arc (u,v*) in A, and any time after that, it
can choose an arbitrary arc in out(u).

Subclaim 7.8.8

Let C' be a connected component of A in [ESSSP| containing a vertex u. If the walk
W contains u, then it contains every arc on the (unique, possibly empty) path from
u to the (unique) cycle in C' (defined as the path from u to the closest vertex on that
cycle).

Proof. Consider the first time the walk visits vertex u. Then, the walk must traverse the
arc (u,v") = €Y, which is the first arc along the path from u to the cycle in C'. We can then
repeat the argument with u replaced by v, considering the first time the walk visits vertex
v*. Continuing this argument until we arrive at a vertex on the cycle proves the claim. ¢

Subclaim 7.8.9

Let C be a connected component of A in containing a vertex u. Suppose a
walk W contains an arc (u,v) with v # v*. Then, the cycles output by the greedy
cycle-finding algorithm contains (1) every arc along the (unique, possibly empty) path
from u to the (unique) cycle in C, and (2) every arc in the (unique) cycle in C' in the

direction given by A.

Proof. For this proof only, imagine that the walk visits one vertex per unit of time, so we
say that the walk reaches a vertex v at time ¢ if v is the i’th vertex of the walk.

We first prove statement (2). Consider the first time 7 that the walk reaches any vertex
in C'; which must occur since vertex u in C' is reached eventually. Then, right after time 1,
the walk will travel along A towards the unique cycle in C, and then travel along C' in the
direction given by A. The greedy cycle-finding algorithm then removes that cycle, proving
statement (2).

We now prove statement (1). If u is inside the cycle of C, then the path is empty and
there is nothing to prove. Otherwise, let (u/,v") be an arbitrary arc along this (nonempty)
path. We first show that (u/,v') is traveled at least once in the walk. For this proof only, for
two vertices u, v in C (possibly u = v), let P(u,v) be the (possibly empty) path in A from
u to v (inclusive), which will always exist and be unique when we use it. Consider the first
time ¢ that the walk reaches any vertex v in P(u,u) (possibly u or u’). Since the walk visits
vertex u at least once, this ‘first time ¢ must occur. Then, right after time 7, the walk must
travel along P(v, '), and then along arc (u/,v’). Let the walk reach vertex v’ at time ¢’ (so
it reaches v at time i’ + 1).

We now show that this first traversal of arc (v/,v") at time ¢’ is indeed added to a cycle
by the greedy algorithm. Define v as before, and consider the next time j the walk reaches
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Figure 7.4: The two cases of statement (1) from Subclaim [7.8.9, The curved paths that do
not align with the horizontal edges are arbitrary paths in other parts of the graph. The red
dashed line marks the cycle for the first case, and the green dotted line marks the cycle for
the second case.

any vertex = in P(u,v). Then, right after time j, the walk must travel along P(z,v). Since
the walk contains an arc (u,v) with v # v*, vertex u is reached at least twice, so this ‘next
time 7’ must occur (we need at least twice in case v = u). By time j, one of two things can

happen (see Figure [7.4):

1. The walk returns to some vertex y in P(v, ') before time j (but after time i’ 4 1). Let
time j' € (¢/+1, j) be the first such return, and let y be the vertex returned to. Before
time j', all vertices in P(v,u’) were visited exactly once, so either all arcs in P(v,v’)
were added to the same cycle before time j, or none of them were added to any cycle
before time j. In the former case, the path P(v,v’) includes arc (u',v"), so we are done.
In the latter case, we obtain the cycle consisting of the path P(y,v’), followed by the
(remaining) arcs in the walk from time ¢’ 4+ 1 to time j’. The greedy algorithm then
adds this cycle, which contains arc (u’,v’).

2. The walk does not return to any vertex in P(v,u') after time i’ + 1 and before time
j. By the same argument as the case above, either all arcs in P(v,v") were added to
the same cycle before time j, or none were added to any cycle before time j. Again,
in the former case, we are done, so suppose the latter. Right after time j, the walk
travels along P(z,v), and no vertex on P(z,v) except possibly v was visited before
time j. Therefore, the next time the walk encloses a cycle (after time j) is when it
travels along P(x,v) and reaches v. At this point, all arcs in P(z,v") (including arc
(u',0")) are added into the cycle that begins and ends at v, so we are done.

Therefore, in both cases, we are done. o
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Subclaim 7.8.10

For each vertex t € V*, let C' be the component in A containing t. We have

length(W;) + 2cycles(W;) > dp.(t,7¢) + de (s, o).

Proof. Define a subwalk of W to be a contiguous subsequence of the walk when it is viewed
as a sequence of vertices. We partition the walk W into subwalks as follows. Start with
the first vertex t of the walk, and let C' be the component of A containing t. Consider the
last vertex v of the walk also inside C. If v = s, then we are done; otherwise, break of the
subwalk from the beginning of the walk to the (last occurrence of) vertex v in the walk, and
recursively apply the procedure on the remaining vertices of the walk.

Let the subwalks be Wy, W, ... Wy in that order, and for ¢ € [k], let u; and v; be the
first and last vertex of W;, and let C; be the component of A containing both u; and wv;.
By construction, all components C; are distinct. For each i € [k — 1], the arc (v;, u;51) in f
is responsible for adding an arc (ve,,ve,,,) in line 15| of weight w(v;, uiy1) + dre, (vi,re,) +
dre,, , (Uis1,7cpy) + ¢(Ci) + ¢(Cipa); let this arc be (vj, ujy ). Consider the path from r¢ to
s’ in G’ consisting of the edges (v;,u; ) for i € [k — 1]. It suffices to show that this path
has length at most length(W;) + 2cycles(W;) — dz. (t, r¢); the distance dg (s, r¢) can only be
smaller. In other words, we want to show that

k—1

(]

(w0, i) + i, (076,) + i, (i1, 7010) + e(C) 4+ (Cinn))
i=1

< length(W;) + 2cycles(W;) — dr,.(t,r¢). (7.23)

For each i € [k — 1], the distance dre, (vi, 7¢,) equals the length of the (possibly empty) path
P, in C; from v; to the (closest vertex on the unique) cycle in C;. Since w;,; is not in C;, we
have u;,1 # v,"™", so by Subclaim , every arc in P; is added to some cycle by the greedy
cycle-finding algorithm. Moreover, by Subclaim every edge in the (unique) cycle in C;
is also added to some cycle. All such arcs mentioned are distinct, so we obtain

k-1

Z (chi (vi,re,) + C(C’i)) < cycles(WW}).

=1

For the distances dr,,  (uiy1,7¢,.,) for i € [k — 1], as well as the distance dr, (u1,7¢,) =
dr,(t,r¢), we charge them to the walk W, directly. For each i € [k], since the walk contains
vertex u; in C;, by Subclaim [7.8.8] it contains all arcs on the path from u; to the cycle

in C;, whose weights sum to exactly chi (u,re,). Finally, we also charge the edge weights
w(vy, uiq1) to the walk Wy, since the walk contains them by construction, and they are edge-
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disjoint from each other and all arcs in any C;. Thus,

E

-1
(w0, i) + i, (07¢,) + i, (i1, 7010) + e(C) 4+ (Cinn))

=1

k—1

k—1

<2 Z (dTCi<Ui7 re;) + C(Q‘)) + Z (w(vi, Ui1) + drg, | (Uit1, TC’HI))
i=1 i=1
k-1 -1

k

< 22 (dTC vi,re;) + ¢(C; )) + Zw Vi, Wit1) Zch ui,re;) — dr, (t,re)
i=1

< 2cyc|es(Wt) + length(W;) — dg. (¢, re),

proving (|7.23)). o

We now resume the proof of Lemma [7.8.6, Multiplying the inequality by b*(¢) for each
t and summing over all ¢ gives

Z bt (t) - length(W;) + 2cycles(WW;) > Z bH(t) - (dr(t,re) + dar(s,7¢)). (7.24)

teV* teV*

Consider routing, for each component C' of A and vertex ¢ in C, b"(¢) amount of flow
along the shortest path from s’ to ve in G'. By construction of the demand vector ¥, this is
a transshipment flow that satisfies demands ¢’. Therefore, the optimum transshipment cost
opt’ can only be smaller (in fact, it is equal), and we obtain

Opt < Z b+ dG/ S 7’0)

tev
Together with (7.24), this concludes (7.22)), and hence Lemma [7.8.6] O

Corollary 7.8.11

Over the randomness of [ESSSP| (in particular, the random choices on line (3)),

Z bt (t) - dr.(u,7c) + opt’| < ]18f+2e opt.

teVv*

Proof. We apply Lemma by taking the expectation of (7.21)) over the randomness on
line [3) which effectively removes the conditioning by F, and obtain

> " b*(t) - Eflength(W})] + 2 - opt > E[b* () - dr.(u, r¢) + opt'].

teV*

This, along with >, .. b*(t) - E[length(W})] < ]laffrom Claim [7.8.4} finishes the proof. [J
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Define 7" and T as in For each component C' of A and each vertex v in C, we
have dr(s,v) < dp (s, ve) + dr, (t,7¢).

Proof. Follows easily by construction (lines [20] to , so proof is omitted. O]

Claim 7.8.13

Over the entire randomness of [ESSSP| (including the recursion at line [18)), we have

E > bH(t) - dr(s t] (1 + 3¢)opt.

teV

Proof. By Claim we have b*(t) =0 for all t € V'\ V*, so it suffices to prove

Z bt (t) - dp(s,t) ] < a1+ 3¢)opt.

teV*

By recursion, we have the guarantee

Z b ~drpi (s vc)] < aopt.

Cwo#s’

For each vertex t, let C; be the component of A containing ¢. We have

Zb+ - dp(s,v)

Clm.[T.812

D BN (1) - dro(' ve,) + dig, (¢ rct))]

teV* teV*
—E Z b (t) - dpi (s, ve,) | +E Z b (t) - dr, (t,7c,)
tEV* _ tEV* .
=E| Y V. -dp(sve)| +E | b(t) - dr, (tre,)
LC:ve#s’ | LteV* J
< OéE[Opt/] +E Z b+(t) ’ ch75 <t7 TCt)]
teV*

Z b (t) ) dTC’t (t’ TC’t)] )

tevs
Cor.[LET] "

< « (]18]‘4— 2¢ - opt)
< a((1+ €)opt + 2¢ - opt) = (1 + 3¢)opt,

<a (]E[opt’] +E
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where the last inequality uses that f is a (1 + e)-approximate transshipment solution. O

7.8.1 Parallelizing the Expected SSSP Algorithm

In this section, we parallelize the algorithm by removing its sequential recursive calls
which would, if left unchecked, blow up the parallel running time of the algorithm. This
is because every time calls itself, it requires a transshipment flow of a new recursive
graph, which in turn requires an ¢;-embedding of it.

Our solution is to avoid computing an ¢;-embedding (from scratch) at each step of the
recursion. With an /;-embedding at hand for a given recursive graph, we can compute the
transshipment flow without any recursion by invoking Corollary Naively, one might
hope that a subset of the original vectors of the ¢;-embedding automatically produces an
(1-embedding for the recursive graph instance. This is not true in general, but we can force
it to happen at a cost: we add a set of virtual edges to the recursive graph instance (which
may change its metric) that come from a spanner of the ¢;-metric on a subset of the original
vectors. These edges may not exist in the original graph G, so the shortest path tree T of that
instance may include edges that do not correspond to edges in the original graph. Unraveling
the recursion, the final tree 7" may also contain virtual edges not originally in G. However,
we ensure that these virtual edges do not change the metric of the original graph GG. Hence,
while the tree T' is not a spanning SSSP tree, it still fulfills its purpose when it is called in the
algorithm of Section 7.9} (If a spanning SSSP tree is explicitly required for the
final output of the original graph instance, then can be called in once on
the initial recursion loop, and called on every recursive instance of

afterwards.)

Lemma 7.8.14

Let G = (V, E) be a connected graph with n vertices and m edges with aspect ratio
poly(n), let € > 0 be a parameter. Given graph G, a source s € V, and an /(;-
embedding of it into O(logn) dimensions with distortion polylog(n), we can compute,
in O(m) work and polylog(n) time, a set E* of virtual edges supported on V and a
tree T'C E U ET such that
1. For all edges (u,v) € ET, w(u,v) > dg(u,V), i.e., the edges ET do not change
the metric of G

2. T is a (1 + €)-approximate expected s-SSSP tree on G U E*

We now present the recursive algorithm that proves Lemma[7.8.14] Let the original graph
be Gy and the current recursive instance be G, and let n and m always indicate the number
of vertices and edges of the original graph G,. We highlight in blue all modifications of
[ESSSP| that we make.

One notable difference is that we always enforce the recursive vertex set VV/ to be a subset
of the current vertex set V. This allows us to compare the metric of the new graph G’ to
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Algorithm 13 [ESSSP-Rec(G = (V, E),{y, € RF : v € V},5,b, (1 + 3¢)a)

Global variables: Gy = (VO,EO) is the original input graph; {z, € RO0e™ : ¢ € V} is an
initial /;-embedding of Gy with distortion D = polylog(n) given as input

Assumption: k = O(log”n) and {y,} satisfies

Sde(;(u v) < 1y, — 9y < (D +6) - dg(u,v)

for all u,v € V; demand vector b satisfies by > 0 and by <0 for allt € V' \ s

1:

{8kD - y,} is an ¢1-embedding of G with distortion 8kD(D + 6) = polylog(n). Apply
dimension reduction to obtain an f;-embedding of G into O(logn) dimensions instead
of O(log”n), with a slightly worse but still polylog(n) distortion (see proof of Theo-
rem [7.4.0). Using it and Corollary [7.3.3] compute a (1 + €)-approximate transshipment
f on G with demand vector b

Initialize the digraph A« 1)

Every vertex u € V \ s with in(u) #  independently samples a random neighbor
v € out(u) with probability f(u,v)/fou(u) and adds arc (u,v) to

Add a self-loop (s, s) of zero weight to

5. Let A be the undirected version of

Initialize G’ < ((),0) as an empty undirected graph > Graph to be recursed on, with
< n/2 vertices
Initialize b’ as an empty vector > Demands to be recursed on

8: for each connected component C' of A do

9:
10:

11:
12:

13:
14:
15:
16:

17:
18:
19:

20:

21:

22:

23:

24
25:

c(C) <+ total weight of edges in the (unique) cycle in C' (possibly the self-loop (s, s))

Let T be the graph C with its (unique) cycle contracted into a single vertex ro >
Tc is a tree

Let vc € V be an arbitrary vertex on the cycle in C'

Add the vertex v to G, and set demand 0], , < Zvev(c) b,

for each edge (u,u’) in £ do

Let C and C' be the connected components of A containing u and u’, respectively

if C # C’ then

Add an edge between v and ver with weight w(u, u') +dr, (u, 7¢) +dg,, (W', 7o) +

c(C) + e(C)
Note that s = ve,, where Cy is the component of A containing s
Collapse parallel edges of G’ by only keeping the parallel edge with the smallest weight
Call Lemma on the original ¢;-embedding vectors {x, : v € V'}, returning a set
of vectors {y, € ROUs*m) ., € V'} that satisfy
Call Lemma on {y, : v € V'}, returning a set Et of edges supported on V' that
satisfy both conditions of Lemma
Recursively call [ESSSP-Rec|G’ U E*, {y/ : v € V'},s,V/, ), obtaining an a-approximate
expected SSSP tree T" of G' U ET
Initialize T' <+ 0 > The expected SSSP tree
for each edge (v,v') in "N E do

Let (u,u’) € E be the edge responsible for adding edge (v,v’) to G’

Add edge (u,u’) to T > Continued on next page
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Algorithm 13 [ESSSP-Rec|G = (V, E), s,b, (1 + 3¢)a), continued

26: for each edge (v,v") in 7"\ E do

27: Let (u,u’) € E be the edge responsible for adding edge (v,v’) to G’

28: Let C and C' be the connected components of A containing u and u’, respectively

29: Add edge (v,v") to T' with weight w(v,v") — ¢(C) — ¢(C") > These edges are not in
the original edge set Ej

30: for each connected component C' of A do

31 Remove an arbitrary edge from the (unique) cycle inside C', and add the resulting
tree to T'

32: return T

the current graph GG in a more direct way.

Each edge (v, ver) added on line [16] has weight at least dg(ve, ver).

Proof. Let (u,u') € E be the edge responsible for adding the edge (ve,ver). We construct
a path in G from ve to ver whose distance is at most w(u, u') + dr. (u,r¢) + dr,, (W', 7¢r) +
c(C) + ¢(C"). By construction of component C, there is a path inside C' from ve to u of
length at most ¢(C') + dr, (u, r¢). Similarly, there is a path inside C’ from u' to ver of length
at most ¢(C’) +dr,, (v',r¢s). Adding the edge (u, ') completes the path from ve to ver. O

Lemma 7.8.16

Given the vectors {z,}, we can compute, in O(m) work and polylog(n) time, a set of
vectors {y, € ROW0g* ) . 4 ¢ Y/ } such that w.h.p., for all vertices ve, ver € V7,

12y, — Ty, +2(c(C) + ¢(C")) < Loy — Yo < Ly, — Ty, +6(c(C) + ¢(C")).
(7.25)

Proof. We introduce O(log?n) new coordinates, indexed by (i,j) € Z x [s]. For a given
component C' of A at this instance, initialize y,, = z,, without the new coordinates. Let
i € Z be the integer i such that ¢(C) € [2¢,2"1), which can take one of O(logn) values in Z.
For each j € [s], let the coordinate at index (7, j) take value £5-2'/s, each with probability
1/2. This concludes the construction of y,..

Fix vertices ve, vor € V', Let 4,7 be the integers such that ¢(C) € [2¢,277!) and ¢(C") €
[27,27*1). If i # ¢, then by construction, the ¢; distance incurred by the additional O(log®n)
coordinates is exactly 5 - 2° + 5 - 2. Moreover, by the input guarantee of {z, : v € V},

da, (ve,ver) < 12y, — Ty, < D - da(ve, ver).
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Therefore,

Ly — Ty, + 2+ (c(C) +¢(C)) < Ly — 3y, +2- 277+ 2.2
< 1wy, — Ty, +5-20+5-27
= LWoe — Yoo (7.26)
< 1y — Ty, +6-20+6-27
< 12y, — Ty, + 6 (c(O) +¢(C)),

as promised. Now suppose that i = ¢’. Then, for each of the s coordinates (i, j), there is a
1/2 probability of contributing 0 to 1yu, — u..,, and a 1/2 probability of contributing 6-2'/s.
Since s = O(logn), by a simple Chernoff bound, the probability that the total contribution
of these s coordinates is in [4 - 2°/s,6 - 2/s] is at least 1 — 1/poly(n). A similar bound to
(7.26)) proves the claim. O

Lemma 7.8.17

Given vectors {y, € RO’ ™} satisfying (7.25)), we can compute, in O(|E|) work and
polylog(n) time, a set E™ of edges supported on V' C V such that
1. For all edges (ve,ver) € ET, wp+(vo,ver) > da, (ve, ver) + 2(c(C) + ¢(C")),

2. For all vertices u,v € V/,

1
Sk_D ~deugt (%U) STy — Yo < (D + 6) : dG'uE+<U> U)-

Proof. Let H be an (8kD)-spanner of the (complete) ¢;-metric induced by the vectors {y, :
v € V'}. That is, for all u,v € V7,

1y, — yo < dg(u,v) < 8kD - 1y, — Y.

We show later how to compute it efficiently, but for now, assume we have computed such a
graph H. We set the edges E™ as simply the edges of H (with the same weights).
Every edge (vo,ver) € ET satisfies

WEg+ (Uc,v(j/) > dH(UCa UC’) > ILyvc — Yoo > ]l'rvc — Ty + 2(0(0) + C(Cl))’

and 1z, — 2y, > dg,(ve, ver) since {z,} is an (1-embedding for Gy, fulfilling condition (1).
For each pair of vertices u,v € V',

darup+ (u,v) < dg(u,v) < 8kD - 1y, — Y,
fulfilling the lower bound in condition (2).
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For the upper bound in condition (2), it suffices to show that, for all edges (v, ver) €
E'UET,
ﬂyu — Yy < (D + 6) s WqrUE+ (U,U).
If (ve,ver) € ET, then

W — Yoo < du(ve,ver) < wh(ve, ver) = waue+ (ve, ver).

Otherwise, if (v, ver) € E’, then for the edge (u,u') € E with u € C and v’ € C’ responsible
for this edge,

(7-20)
Woe = Yoo < Ly — Ty, +6(c(C) + ¢(C))
< D -dg,(ve,ver) + 6we (ve, ver)

(7.27)
< D- dg(vc, UC/) + 6"CUG’(UCa UC’)

Clm[T8TH]
< D weg(ve,ver) + 6we (ve, ver),

assuming the following inequality holds:
da(u,v) > dg, (u,v) Yu,v € V. (7.27)

To prove ([7.27), we assume by induction (on the recursive structure of that
da(u,v) > dg,(u,v) for all u,v € V. By Claim [7.8.15, the edges (u,v) added to G’ on

line |16 have weight at least dg(u,v) > dg,(u,v). Moreover, since condition (1) of the lemma
is satisfied, we have w(u,v) > dg,(u,v) for all edges (u,v) € ET as well. It follows that
dg,(u,v) < deup+(u,v) for all u,v € V') fulfilling the inequality as well as completing
the induction. This concludes the upper bound of condition (2), as well as the proof of
Lemma modulo the construction of the spanner H.

Finally, we show how to compute the spanner H of the ¢;-metric induced by the vectors
{y, € R* : v € V}. We use a randomly shifted grid approach similar to the one in Algo-
rithm |§| Assume without loss of generality (by scaling and rounding) that all coordinates
are positive integers with magnitude at most M = poly(n). We repeat the following process
O(logn) times, computing a graph H; on trial j. For each W a power of two in the range
[1,8kM], choose independent, uniformly random real numbers r1,...,r, € [0,V), and de-
clare equivalence classes on the vertices where two vertices u,v € V' are in the same class if
| (Yu)i + 7i]w = | (yw): + 7iJw for all coordinates i € [k]. For each equivalence class U C V,
select an arbitrary vertex u € U as its representative, and for all v € U \ {u}, add an edge
(u,v) to H; of weight 1y, —y,. This concludes the construction of graph H;, which has
O(|V|log |V|) edges, since each value of W adds at most |V| edges. The spanner H is the
union of all graphs H; and has size O(|V|log |V |logn).

Since all added edges (u, v) have weight 1y, — vy, > dg(u,v), distances in the final spanner
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H are at least the distances in G. To show that distances in H are not stretched too far,
we show that for any vertices w,v € V, with constant probability, each graph H; satisfies
dp;(u,v) < 8kD - dg(u,v). Then, since there are ©(logn) graphs Hj, the probability that
we do not have dy(u,v) < dg,(u,v) < 8kD - dg(u,v) for any j is 1/poly(n), as promised.
Let W be the smallest power of two greater than or equal to 21y, —y,. Note that
1y, — y, < 2kM, so such a W always exists. It is not hard to show that, for each coordinate
€ [k], the probability that [ (y.); +7iJw = [(yu)i +ri]w is exactly 1 —|(yu — o )il /W > 1/2.
Therefore, the probability that |(y.): +7:Jw = |(yu)i + 7i]w for all i is

H(l . ) H ( Wyv>i|) - (_22 [ I}y»zw)
= exp (—ZW) > exp(—1),

which is a constant. Therefore, with at least constant probability, u and v belong to the
same equivalence class for W. In this case, since all vertices in this equivalence class have
their vectors in a cube of side length W, we either added the edge (u,v) of weight 1y, — y, <
D - dg(u,v), or we selected some vertex v' and added the edges (u,v") and (v,v’) of total
weight at most

Ty, — Yo + 1y, — Y < 2EW <2k -41y, —y, <2k -4- D - dg(u,v).

Thus, H is a 8kD-spanner w.h.p. O

We now argue that, for the input graph G, source s € Vj, demand vector by, and an £-
embedding {z, € RO1¢™ : 4 € V} of G with distortion D, [ESSSP-Red|(Go, {2}, 5, bo, (1 +
3e€)l827) returns a (1 + 3¢)°82"-approximate expected s-SSSP tree. We will follow the argu-
ments from Section [7.8 almost line-by-line; the only changes we will highlight in blue. Define
V* b, 8, fon the input graph G = (V, F') and demands b identically as in Section As
before, define opt = opt(b).

Claim 7.8.18: Restatement of Claim 7.8.12

Define 7" and T as in [ESSSP-Rec| For each component C of A and each vertex v in
C, we have dr(s,v) < dp(s',vc) + dr.(t, rc).

Proof. Follows easily by construction (lines 23] to , so proof is omitted. Observe that the
extra terms — ¢(C') — ¢(C”) in line [29] are necessary here. O
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Corollary 7.8.19: Restatement of Corollary 7.8.11

Over the randomness of [ESSSP-Rec| (in particular, the random choices on line |3)),

E | > 67(t) - dr.(u,re) + optaps (V) | < 1C f +2¢ - opt.

tev*

The optimum can only decrease with the addition of edges E™ to G’, so we have

optaryp+ (B') < opte(B'). (7.28)

Claim 7.8.20: Restatement of Claim 7.8.13

Over the entire randomness of ESSSP-Rec]| (including the recursion at line , we have

teV

Zb+ ) - dr(s,t) ] < a1 + 3¢€)opt.

Proof. We follow the proof of Claim [7.8.13] By Claim we have b(t) = 0 for all
t € V'\ V*, so it suffices to prove

Z bt (t) - dp(s,t) ] < a1+ 3¢)opt.
tev

By recursion, we have the guarantee

Z b ~dpi (8 vo)] < aopt.

Cvo#s’

For each vertex t, let C; be the component of A containing t. We have

Clm.[T.818
E > b dev)] < E

teVv*

Z (b+ (t) ) dT'(S/7 Uct) + dTCt (ta TCt))

teVv*

—E Zzﬁ cdpi(s've) | +E | Y bE(E) - day, (tre,)

tGV* J LteV* J

=E| Y b, -dp(sve)| +E | bH(t) - dr, (L re,)

LC:vo#s’ LteV*
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< aEfoptap+ (V)] + E

Z b+ ch t Tct)]

teVv*
(7.28)
< aElopty (V)] +E Z br(t) - dr, t'r’ct)]
teVv*
<a (E[optG,(b’)] +E [ Dbt - dr, (¢, WD
teV*

Cor.[Z8 19
< (18 Fa2e opt)
< a((1 4+ €)opt + 2¢ - opt)
= (1 + 3¢)opt,
where the last inequality uses that f is a (1 + €)-approximate transshipment solution. n

Thus, by unraveling the recursion and repeatedly applying Claim the algorithm
ESSSP-Rec| computes a tree T' for Gy, possibly with virtual edges not in Gy, such that

Zb+ ) - drp(s,t) ] < (1 + 3e)'e2"0opt.
tev

It remains to show that 7' is a “valid” approximate expected SSSP tree in the following sense:
the virtual edges added to T" do not change the metric on GGy. This property is sufficient
when the expected SSSP algorithm is used in

For each vertex v € Vj, consider the recursive instances with v € V; let C;(v) be the
components containing v on previous levels of recursion, with C; as the value at recursion
level i. We will need the following quick claim:

Claim 7.8.21

Consider a recursion level ¢ with input graph G = (V, E). For all vertices v € V| the
lengths ¢(C;(v)) of the cycles satisfy ¢(Cj+1(v)) > 2¢(Cj(v)) for all j < i.

Proof. If v = s, then it holds because ¢(Cj(v)) = 0 for all j, so assume that v # s. For a
fixed recursion level j, by construction, every edge adjacent to v has weight at least ¢(C;(v))
in the graph G’ at that level. The cycle C;;;(v) must contain at least two such edges, so its
total length is at least 2¢(C;(v)). O

For each virtual edge (u,v) in T, there exists some recursion level ¢ and some edge
(u,v) € ET at that level of weight

i

wp+ (u,0) = wr(u,0) + Y (e(C(w)) + (C;(v))).
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By Claim [7.8.21}, the sequence ¢(Cy(u)), c(Ci(u)), ..., c(Ci(u)) = ¢(C) has each term at least
double the previous, which means that two times the last term is at least the sum of all
terms:

2¢(C) = 2(C5(w) = Y e(Ci(w).

Similarly,
2(C") = 2e(C,(0)) 2 3 elCy(0).

By condition (1) of Lemma [7.8.17]
wpe (,0) > day (1, 0) + 2(c(C) + ().

Combining these inequalities gives

i

wr(u,v) = wes (u,0) = Y (e(C(w)) + ¢(C5(0))) = wp (u,0) = 2(e(C) +¢(C")) = day (u, ),

J=0

as claimed.

7.9 Sampling a Primal Tree

In this section, we prove Theorem . by reducing the problems of computing a (1 + €)-
approximate SSSP tree and potential to the approximate transshipment problem, and then
using the expected SSSP tree subroutine of Section [7.8.1] Most of these ideas
originate from [14], and we adapt their ideas and present them here for completeness. In
particular, we do not claim any novelty in this section.

Theorem: Restatement of Theorem 7.3.7

Let G = (V, E) be a graph with n vertices and m edges, and let € > 0 be a parameter.
Given graph G, a source s € V', and an ¢;-embedding of it into O(logn) dimensions
with distortion polylog(n), we can compute a (1 + €)-approximate SSSP tree and
potential in additional O(m/e?) work and O(1/€®) time.

We now briefly describe our algorithm for Theorem [7.3.7 First, we run the expected
SSSP algorithm with demands ), (1, — L;), obtaining distances that are near-
optimal in ezxpectation in the two different ways. Of course, what we need is that all distances
are near-optimal. This is where the potential ¢ is useful: using it, we can approximately
tell which vertices have near-optimal distances. Then, among the vertices V' C V' \ s
whose distances are not near-optimal, we then compute another transshipment instance
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with demands ) .., (1, — 1,) and repeat the process. As long as the set of remaining
vertices V' drops by a constant factor each round in expectation, we only require O(logn)
rounds w.h.p.

To construct the potential, our strategy is simple: we simply take the coordinate-wise
maximum of all potentials ¢ found over the iterations (assuming ¢(s) = 0 always). For each
vertex v € V' \ s, since at least one iteration computes a near-optimal distance for v, the
corresponding potential is also near-optimal.

Constructing the specific SSSP tree requires a little more care. We now describe our
algorithm in pseudocode below.

Algorithm 14 [TS-to-SSSP (V,E), B € (0,1],{z, € ROUen) .y ¢ V1)
Assumption: {z, € ]Ro(log”) NS V} is an ¢;-embedding of G with distortion polylog(n)

1: Initialize V' < V' \ s > V" is the set of vertices whose distances still need to be computed

2: Initialize d* : V'\ s = RU oo as d*(v) = oo everywhere > d* tracks the best distance
found for each vertex v

3: Initialize p* : V' \ s - VU{L} as p*(v) = L everywhere > p* is the “parent” function,
used to construct the final SSSP tree

4: Initialize ¢* : V' \ s = R as ¢*(v) = 0 everywhere > ¢* tracks the best potential found
for each vertex v

5. while V' # () do

. With the ¢;-embedding {z, : v € V}, call Corollary on demands Y, (1, — 1)

to obtain (1 + €/10)-approximate flow-potential pair (f,¢), where f is an acyclic flow
and ¢(s) =

7 Set demands b < > .. (1, — 1) for this iteration

8:  With the ¢;-embedding {x, : v € V}, call (G, {z, 10 €V}, 5,0,0(55))
to obtain a SSSP tree T with E [>°, .y dr(s,v)] < (1+ 35)opt > Satisfies the recursion
in (7-13)

9: Compute distances dr(s,v) for all v € V’

10: for each vertex v € V' in parallel do

11: Let p(v) be the second-to-last vertex on the path from s to v in T’

12: if dr(s,v) < d*(v) then

13: Update d*(v) < dr(s,v)

14: Update p*(v) + p(v)

15: Update ¢*(v) < max{¢*(v), ¢(v)}

16: if dr(s,v) < (1+¢€)¢, then

17: Remove v from V'

18: Initialize T* < () > T™ is the final SSSP tree that we compute

19: for each vertex v € V' \ s do
20: Add edge (v, p*(v)) to T*
21: return SSSP tree T* and potential ¢* (augmented with ¢*(s) := 0)
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If TS-to-SSSP| finishes, then the potential ¢* that it returns is a (1 + €)-approximate
s-SSSP potential, and the returned T* is a (1 + €)-approximate SSSP tree.

Proof. essentially takes the coordinate-wise maximum over all potentials ¢ that
Algorithm A computes over the iterations. For each vertex v € V'\ s, since it was removed
from V' at some point, some potential ¢ computed satisfies (1 + €)¢, > dr(s,v) > d(s,v)
(line [16), so the final potential ¢* also satisfies (1 + €)¢} > d(s,v). Since each potential
¢ satisfies |p(u) — ¢(v)| < w(u,v) or each edge (u,v), by Observation so does the
coordinate-wise maximum ¢*. Therefore, ¢* is an SSSP potential.

Next, we show that the graph T returned is indeed a tree. Observe the following invari-
ant: for each vertex v € V', whenever p*(v) # L, we have d*(p*(v)) < d*(v). This is because
whenever d*(v) is updated to dr(s,v) on line [13] we must have dr (s, p*(v)) < dr(s,v) for
that tree T, so d*(p*(v)) would be updated as well if it was still at least dr(s,v). Therefore,
the edges (v, p*(v)) at the end of the while loop must also satisfy d*(p*(v)) < d*(v), so the
edges are acyclic. Since there are n — 1 edges total, T* is a tree. Finally, to show that T™ is
a (14 ¢)-approximate SSSP tree, we show that for each vertex v € V', dp«(s,v) < d*(v). To
see that this is sufficient, observe that since every vertex v € V'\ s was removed from V', we
have dr(s,v) < (14 €)¢, < (1+ €)d(s,v) at some point (line [16), so on this iteration, d*(v)
would be updated to at most (1 + €)d(s,v).

We prove by induction on the ordering of d*(v) (from smallest to largest) that dp«(s,v) <
d*(v). If p*(v) = s, then since d*(v) and p*(v) are updated at the same time (lines[13|and[14),
the value d*(s) = dr(s,v) = w(s,v) cannot be changed after p*(v) was set to s. Therefore,
dr«(s,v) = d*(v). Otherwise, suppose that p*(v) = u # s. Let T' be the tree computed on
the iteration when p*(v) was updated to its final value. We have

(ind.)
dT* (S,’U) = dT* (Sa u) + w(u, U) < d*<u) + w<u7 U) < dT(87 u) + w<u7 U)a

where the (ind.) stands for applying the inductive statement on vertex u. This completes
the induction and the claim. O

For the remainder of this section, we show that the while loop runs for only O(logn)
iterations w.h.p. Consider the following potential function ) . b,d(s,v); we will show that
it drops by a constant factor in expectation on each iteration of the while loop. Since the
graph G has polynomial aspect ratio, the potential function can only decrease by a constant
factor O(logn) times, so the lemma below suffices to finish Theorem [7.3.7]

On each iteration of the while loop (line , the quantity ) .., d(s,v) drops by a
constant factor in expectation.
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Proof. Define d(v) := E[dr(s,v)] over the randomness of on this iteration, which
satisfies Y, d(v) < (14€/10) 3, .y d(s,v) since T is an (1 +€/10)-approximate expected
SSSP tree with demands ) .. (1, — 1,). Since (f, ) is a (1 + ¢/10)-approximate flow-
potential pair, we have 1f < (1 +¢/10) ", .\, byd,. We have, for small enough e,

which implies that

PCOETAES DY

veV! veV!

Observe that for all v € V', d(v) > d(s,v) > ¢y — ¢ps = ¢y, 50 d(v) — ¢, > 0. Let V!

V/
good
be the vertices v € V' with

— €
d(’U) - ¢v S §¢v
By a Markov’s inequality-like argument, we have

> b= % > s (7.29)

veVg’OOd veV/’

otherwise, we would have

Y (d(v) =) = Z (d(v) = ¢0) =

veV’ veV\V!

l\DIm

Z 0> 7D b
VO veV’

good od

a contradiction.

For each v € Vj, 4, by Markov’s inequality on the nonnegative random variable dr(s,v) —
¢, (which has expectation d(v) — ¢, < £¢,), with probability at least 1/2, we have

dr(s,v) — ¢y < €y <= dr(s,v) < (14 €)py,

so vertex v is removed from V'’ with probability at least 1/2. In other words, the contribution
of v to the expected decrease of Y, i d(s,u) is at least ¢,. Since

d(s,v) < dr(s,v) < (1+€)py = ¢u > (1+¢€)d(s,v),

186



!/

ood, the expected decrease

this expected decrease is at least ﬁgﬁv Summing over all v €
of v d(s,u) is at least

! |
Z —d(37 U) Z 71 N Z d(S, U)u
veVg’ood 2(1 T 6) 4(1 + 6) veV!’
which is a constant factor. O

7.10 Omitted Proofs

7.10.1 Proof of Lemma [7.3.5

Lemma: Restatement of Lemma 7.3.5

Given a transshipment instance with graph G' = (V, E) with n vertices and m edges
and an integer demand vector b satisfying |b,| < M for all v € V', we can transform G
into another graph G on n vertices and at most m edges such that G has aspect ratio
at most n*M, and optg(b) < optg(b) < (1 + 1/n?) optg(b). The transformation takes
O(m) work and polylog(n) time.

Proof. Suppose that the demand vector b satisfies b, € {0,1,2,...,n%} for all v € V, for
some constant C. First, compute a minimum spanning tree 7" of the graph G = (V| E )E and
compute the optimal transshipment cost where the input graph is 7" instead, which is easily
done efficiently since T' is a tree. Since 7' is a minimum spanning tree, it is easy to see that
for any vertices u,v € V', we have dg(u,v) < dp(u,v) < (n—1)-dg(u,v), i.e., the stretch of
T is at most (n — 1). Define Z := opt,(b) as the optimal transshipment cost on T'; it follows
that

opte(b) < Z < (n—1)opty(h). (7.30)

To construct é, we start with G and remove all edges of weight more than Z from G,
and then add Z/n“*> weight to each remaining edge in the graph. Clearly, G has aspect
ratio poly(n) and satisfies opt(b) < opts(b). It remains to show that

opts(b) < (1 + %) opte(b). (7.31)

The transshipment problem can be formulated as an uncapacitated minimum cost flow
problem. It is well-known that if the demands of a minimum cost flow problem are integral,
then there exists an optimal flow that is integral. Let f be this integral flow for demands

"This can be computed work-efficiently in Parallel, e.g., with Boruvka’s algorithm.
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b. Then, f cannot carry any flow along any edge with weight more than 7, since if it did,
then it must carry at least 1 flow along that edge, bringing its total cost to more than 7,
contradicting the fact that opt,(b) < optp(b) = Z. It follows that removing edges with
weight more than Z does not affect the optimal transshipment cost.

Since |b,] < nY for all v € V, it is also well-known that the optimal flow f satisfies
|f.] < n® for all e € E. Consider the same flow f on G instead of G; since each edge has its
weight increased by Z/n®*4, the total increase in cost of the flow f on G is

(7.30)
S iht L (2) e L 2P Bl

nC+5 —\2 TLC+5 - nd - n2

ecE

The cost of the optimal flow on G can only be lower, which proves l} [

7.10.2 Proof of Lemma [7.3.12

Lemma: Restatement of Lemma 7.3.12

Let G = (V, E) be a graph with n vertices and m edges, and let A be an algorithm
that inputs any vertex set S C V and outputs a (1 + 1/logn)-approximate S-SSSP
potential of G. Then, there is an algorithm that computes an ¢;-embedding of G into
O(log®n) dimensions with distortion O(log®n) and calls A at most O(log®n) times,
plus O(m) additional work and polylog(n) additional time.

Algorithm 15 L1_embed(G = (V, E))

1: Let N < O(logn), T < [logn], € + 1/logn

2: for independent iteration i =1,2,..., N do

3: fort=1,2,...,T do

4: Sample each vertex in G independently with probability 1/2%; let S be the sampled
set

5: Compute (1 + €)-approximate S-SSSP potential ¢;;(u) of G through algorithm A

6: Extend ¢;; so that ¢;(v) = ¢;4(s) for all v € S, so that ¢;,(v) is now defined for
allv eV

7. For each v € V, output the vector z(v) = (50:1:(v))icivi e € RV*IT as the £;-
embedding of v

Fix two vertices u, v throughout the proof, and define d := d(u, v); we need to show that
w.h.p.,

o) =)l = 57 X 16l - Guloll € |

1€[N],t€[T]

Ollog®n) d}'

The upper bound is easy: by definition of approximate s-SSSP potential, we have |¢;¢(u) —
¢it(v)] < d for all 4,t, so taking the average over all i,t gives 57 >, , [dir(u) — ¢ii(v)| < d.
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To finish Lemma it remains to prove the lower bound, whose proof occupies the rest
of this section.

There is a value of t € [T] such that with probability (1),

| P, (u) — ¢is(v)] > Qed).

For each positive r, define B(u,r) := {w € V : d(w,u) < r} as the vertices within
distance r from w. Similarly, define B(v,r) as the vertices within distance r from v.

There exists a value r € [d/6,d/3] and a (universal) constant C' > 1 such that

|B(u, (1 +2¢)r)| < C|B(v,r)| or |B(v,(1+2€)r)| < C|B(u,r)]|.

Proof. First, we show that such a value r must exist. If not, then we have the chain of
inequalities

d 1 d 1 d
|B(u, (1 + 26)6)| < §|B(U, (1+ 26)26)| < @|B(U7 (1+ 26)36)|

o
Ql~

1 1d

e < a|B(U, (14 2¢) g)|

for L = [log( 190 2] = O(1/¢) = O(logn) (we assume w.l.o.g. that the last expression has u
and not v). For large enough C| this means that

d 1 Ld 1 d Ld
1 < ’B(U,g” < WlB(U,(1+2€> 6)| < E'B(U,(l—FQG) 6)‘ — ’B(U, (1+2€) 6)‘ >n

which is impossible. Therefore, such a value r exists. n

Take the value 7 guaranteed by Claim[7.10.4] and assume w.l.o.g. that |B(u, (14 2¢)r)| <
C|B(v,r)|. Pick t € [T] satisfying 27! < |B(v,r)| < 2!, which also means that |B(u, (1 +
2¢)r)] < O(2"). Suppose we sample each vertex in V' with probability 1/2¢ (line [4)). With
probability ©(1), we sample at least one vertex in B(v,r), and with probability (1), we
sample zero vertices in B(u, (1 + 2¢)r). Moreover, since r + (1 +2¢)r < 3r < 3-d/3 = d, the
two sets B(u, (1 + 2¢)r) and B(v,r) are disjoint, so the two events are independent. Thus,
with probability (1), we have both S N B(v,r) # 0 and S N B(u, (1 4+ 2¢)r) = @, which
implies that d(S,v) < r and d(S,u) > (1 + 2¢)r.

Fix an iteration ¢ € [N], and let us condition on the previous event. Since ¢;; is a
(1 4 €)-approximate S-SSSP potential of G, we have ¢,,(v) — ¢ii(s) < d(S,v) < r by
property (1) of Deﬁnitionand Gip(t) = Pie(s) > Tzd(S,u) > i (142€)r = (14+Q(e))r
by Observation [7.2.9) Thus, [¢;(u) — ¢is(v)] = Q(e) - 7 > Qe) - d/6 = Q(ed).
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Since there are N = O(logn) trials, w.h.p., one of the iterations i € [N] will satisfy
|pit(w) — @i (v)| > Q(ed) for the value of ¢ guaranteed by Lemma [7.10.3] Thus, w.h.p., we
have

Jo(u) - 2(0)], = Zwm — o) 2 pQed) = 5l

concluding Lemma [7.10.3

7.10.3 Proof of Lemma [7.5.10

Lemma: Restatement of Lemma 7.5.10

Given a tree T= (V, E) and a set of sources S C V', we can compute an exact S-SSSP
potential in O(m) work and polylog(n) time.

Proof. It suffices to compute (exact) S-SSSP distances on 7', after which we simply define
¢(v) as the distance to v for each vertex v.

Define a centroid of the tree T as a vertex v € V such that every component of T" — v
has size at most |V|/2. We can compute a centroid r as follows: root the tree 7" arbitrarily,
and for each vertex v, compute the size of the subtree rooted at v; then, let the centroid be
a vertex whose subtree has size at least n/2, but whose children each have subtrees of size
less than n/2. Next, compute the distance dr(r, S) from r to the closest vertex in S, which
can be accomplished by computing SSSP on the tree with r as the source. Now root the
tree T" at r, and for each child vertex v with subtree T;,, construct the following recursive
instance: the tree is T, together with the edge (v,r) of weight dr(r,S) + w(v,r), and the
set S is (V(T,) NS) U {r}. Solve the recursive instances, and for each vertex u € V \ r,
the distance d(u) is the computed distance in the (unique) recursive instance 7, such that
u €T,

It is clear that the above algorithm is correct and can be implemented in O(m) work and
polylog(n) time. O

7.11 Conclusion

In this chapter, we presented a parallel approximate shortest path algorithm that is optimal
up to polylog(n) factors. Unfortunately, the number of polylog(n) factors is prohibitively
large (in the dozens), rendering this algorithm far from practical.

Our result was actually achieved concurrently to the one of Andoni, Stein, and Zhong [9],
who considered the slightly simpler problem of approximate s—t path. Both results used
the same high-level framework and appeared in the same conference (STOC 2020). The
algorithm of Andoni et al. also suffers a large number of polylogarithmic factors, although
for a different reason.
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Lowering the number of logarithmic factors to, say, single digits is an interesting open
problem that will require many new insights. Such an algorithm will almost certainly be
simpler and more natural, and would likely broaden our understanding of the parallel shortest
path problem.
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Chapter 8

Deterministic Expander Decomposition

In this chapter, based on [27, [72], we present the deterministic expander decomposition
routines that we use in preconditioning-based algorithms for other chapters of the thesis.
There are two main variants of expander decompositions that we need, both for weighted,
undirected graphs. The first considers additional custom demands on the vertices, which are
necessary for the deterministic Steiner mincut algorithm of Section [3.3] The second variant
does not require vertex demands, but enforces an additional boundary-linkedness condition
that is necessary for the global mincut application (Chapter @

However, for most of the chapter, we will stick with the standard expander decomposition
setting on unweighted graphs, following the treatment in [27]. At a high level, the algorithm
uses the cut-matching game technique of Khandekar, Rao, and Vazirani [59], originally de-
veloped for the sparsest cut problem. Their original framework is randomized, so instead. we
apply a recursive strategy that reduces the cut-matching game setting to smaller instances
of itself.

We then use unweighted expander decomposition as a subroutine to solving the weighted
setting with custom demands, following the work of [72]. Finally, we augment the weighted
expander decomposition algorithm (with standard demands) to handle the additional
boundary-linkedness condition.

8.1 Background

In its standard form, an (e, ¢)-expander decomposition of an unweighted graph G = (V, E) is
a partition P = {V1, ..., Vi } of the set V of vertices, such that the graph G[V;] is a ¢-expander
(see Definition ) for all 1 <i <k, and Zf_l dc(V;) < evol(G). This decomposition was
introduced in [43], 53] and has been used as a key tool in many applications, including the
ones mentioned in this chapter.

Spielman and Teng [100] provided the first near-linear time algorithm, whose running time
is O(m,/poly(e)), for computing a weak variant of the (e, €2/poly(log n))-expander decompo-
sition, where, instead of ensuring that each resulting graph G[V;] has high conductance, the
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guarantee is that for each such set V; there is some larger set W; of vertices, with V; C W},
such that ®(G[W;]) > €%/poly(logn). This caveat was first removed in [87], who showed
an algorithm for computing an (e, ¢/n°®)-expander decomposition in time O(m!'*°®M) (we
note that [104] provided similar results with somewhat weaker parameters). More recently,
[94] provided an algorithm for computing (e, €/poly(log n))-expander decomposition in time
O(m/e). Unfortunately, all algorithms mentioned above are randomized.

The only previous subquadratic-time deterministic algorithm for computing an expander
decompositions is implicit in [41], running in time O(m!>+°W).

Weighted Graphs with Additional Requirements. Recently, a number of results have
been discovered that require an expander decomposition routine on weighted graphs with
additional requirements. These include the deterministic mincut algorithm of Chapter [6] and
the deterministic Steiner mincut algorithm of Section [3.3] Since the unweighted case remains
the main contribution of this chapter and does not require definitions or techniques from

these weighted variants, we defer their details to Sections and

8.1.1 Our Techniques: Unweighted

Our unweighted deterministic expander decomposition algorithm uses a core subroutine that
we name BalCutPrune, which will become the main focus for most of this chapter.

Definition 8.1.1: BalCutPrune problem

The input to the a-approximate BalCutPrune problem is a graph G = (V, E), a conduc-
tance parameter 0 < ¢ < 1, and an approximation factor . The goal is to compute a
cut (A, B) in G, with |Eq(A, B)| < a¢ - vol(G), such that one of the following holds:
either

1. (Cut) volg(A),volg(B) > vol(G)/3; or

2. (Prune) volg(A) > vol(G)/2, and graph G[A] has conductance at least ¢.

BalCutPrune is a simpler problem to study, and the reduction from expander decompo-
sition to BalCutPrune is straightforward: start with the original graph, and iteratively call
BalCutPrune on any cluster that is not a certified ¢-expander to split it into two smaller
clusters. We formalize this algorithm and reduction in Section [3.6]

The main technical result of this chapter is a deterministic algorithm for BalCutPrune.

Theorem 8.1.2

There is a deterministic algorithm, that, given a graph G with m edges, and
parameters ¢ € (0,1], 1 < r < O(logn), and a = (logm)”, computes
a solution to the a-approximate BalCutPrune problem instance (G,¢) in time
O <m1+0(1/r)+o(1) . (log m)O(r2)/€2>'
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Our algorithm for the proof of Theorem is based on the cut-matching game frame-
work that was introduced by Khandekar, Rao and Vazirani [59], and has been used in numer-
ous algorithms for computing sparse cuts [41], 59, 87, 94] and beyond (e.g. [21], 22, 26], OT]).
Intuitively, the cut-matching game consists of two algorithms: one algorithm, called the cut
player, needs to compute a balanced cut of a given graph that has a small value, if such
a cut exists. The second algorithm, called the matching player, needs to solve (possibly
approximately) a single-commodity maximum flow / minimum cut problem. A combination
of these two algorithms is then used in order to compute a sparse cut in the input graph, or
to certify that no such cut exists. Unfortunately, all current algorithms for the cut player
are randomized. Our main technical contribution is an efficient deterministic algorithm that
implements the cut player. The algorithm itself is recursive, and proceeds by recursively
running many cut-matching games in parallel, on much smaller graphs. This requires us
to adapt the algorithm of the matching player, so that it solves a somewhat harder multi-
commodity flow problem. We now provide more details on the cut-matching game and on
our implementation of it.

Overview of the Cut-Matching Game. We start with a high-level overview of a variant
of the cut-matching game, due to Khandekar et al. [58]. We say that a graph W is a -
expander if it has no cut of sparsity less than . We will informally say that W is an
expander if it is a 1-expander for some 1) = 1/n°Y. Given a graph G = (V, E), the goal
of the cut-matching game is to either find a balanced and sparse cut in GG, or to embed an
expander W = (V| E’) (called a witness) into G; note that W and G are defined over the
same vertex set. The embedding of W into G needs to map every edge e of W to a path
P, in G connecting the endpoints of e. The congestion of this embedding is the maximum
number of paths in {P. | e € E(W)} that share a single edge of G. We require that the
congestion of the resulting embedding is low. Such an embedding serves as a certificate that
there is no sparse balanced cut in G. This follows from the fact that, if W is a ¢-expander,
and it has a low-congestion embedding into another graph G, then G itself is a ¢’-expander,
where ¢/’ depends on 1 and on the congestion of the embedding. The algorithm proceeds via
an interaction between two algorithms, the cut player, and the matching player, and consists
of O(logn) rounds.

At the beginning of every round, we are given a graph W whose vertex set is V', and its
embedding into G; at the beginning of the first round, W contains the set V' of vertices and
no edges. In every round, the cut player either:

(C1) “cuts W”, by finding a balanced sparse cut S in W; or
(C2) “certifies W” by announcing that W is an expander.

If W is certified (Item|(C2))), then we have constructed the desired embedding of an expander
into GG, so we can terminate the algorithm and certify that G has no balanced sparse cut. If

a cut S is found in W (Item |(C1)]), then we invoke the matching player, who either:
(M1) “matches W”, by adding to W a large matching M C S x (V'\ S) that can be embedded
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into G with low congestion; or
(M2) “cuts G”, by finding a balanced sparse cut T in G (the cut 7' is intuitively what prevents
the matching player from embedding a large matching M C S x (V' \ S) into G).

If a sparse balanced cut 7T is found in graph G (Item |(M2)), then we return this cut and
terminate the algorithm. Otherwise, the game continues to the next round. It was shown in
[58] that the algorithm must terminate after ©(logn) rounds.

In the original cut-matching game by Khandekar, Rao and Vazirani [59], the matching
player was implemented by an algorithm that computes a single-commodity maximum flow
/ minimum cut. The algorithm for the cut player was defined somewhat differently, in that
in the case of Item , the cut that it produced was not necessarily sparse, but it still
had some useful properties, which guaranteed that the algorithm terminates after O(log®n)
iterations. In order to implement the cut player, the algorithm of [59] (implicitly) considers n
vectors of dimension n each, that represent the probability distributions of random walks on
the witness graph, starting from different vertices of G, and then uses a random projection of
these vectors in order to construct the balanced cut. The algorithm exploits the properties
of the witness graph in order to compute these projections efficiently, without explicitly
constructing these vectors, which would be too time consuming. Previous work (see, e.g., [25],
94]) implies that one can use algorithms for computing mazimal flows instead of maximum
flows in order to implement the matching player in near-linear time deterministically, if the
target parameters 1/¢,a < n°)). This still left open the question: can the cut player be
implemented via a deterministic and efficient algorithm?

A natural strategy for derandomizing the algorithm of [59] for the cut player is to avoid
the random projection of the vectors. In a previous work of a subset of the authors with
Yingchareonthawornchai [41], this idea was used to develop a fast PageRank-based algorithm
for the cut player, that can be viewed as a derandomization of the algorithm of Andersen,
Chung and Lang for balanced sparse cut [§]. Unfortunately, it appears that this technique
cannot lead to an algorithm whose running time is below ©(n?): if we cannot use random
projections, then we need to deal with n vectors of dimension n each when implementing the
cut player, and so the running time of 2(n?) seems inevitable. In this chapter, we implement
the cut player in a completely different way from the previously used approaches, by solving
the balanced sparse cut problem recursively.

We start by observing that, in order to implement the cut player via the approach of [58],
it is sufficient to provide an algorithm for computing a balanced sparse cut on the witness
graph W; in fact, it is not hard to see that it is sufficient to solve this problem approximately.
However, this leads us to a chicken-and-egg situation, where, in order to solve the BalCutPrune
problem on the input graph G, we need to solve the BalCutPrune problem on the witness
graph W. While graph W is guaranteed to be quite sparse (with maximum vertex degree
O(logn)), it is not clear that solving the BalCutPrune problem on this graph is much easier.

This motivates our recursive approach, in which, in order to solve the BalCutPrune prob-
lem on the witness graph W, we run a large number of cut-matching games in it simultane-
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ously, each of which has a separate witness graph, containing significantly fewer vertices. It
is then sufficient to solve the BalCutPrune problem on each of the resulting, much smaller,
witness graphs. We prove the following theorem that provides a deterministic algorithm for
the cut player via this recursive approach.

Theorem 8.1.3

There is an universal constant Ny, and a deterministic algorithm, that we call CU-
TORCERTIFY, that, given an n-vertex graph G = (V, F) with maximum vertex degree
O(logn), and a parameter r > 1, such that n'/" > N, returns one of the following:

e cither a cut (A4, B) in G with |A|,|B| > n/4 and |Eg(A, B)| < n/100; or
e asubset S C V of at least n/2 vertices, such that W(G[S]) > 1/1og?" n.
The running time of the algorithm is O (nHO(l/T) - (log n)O(T2)>.

We note that a somewhat similar recursive approach was used before, e.g., in Madry’s
construction of j-trees [78]|, and in the recursive construction of short cycle decomposi-
tions [24] [76]. In fact, [41] use Madry’s j-trees to solve BalCutPrune by running cut-matching
games on graphs containing fewer and fewer nodes, obtaining an (m!'®+°("))-time algorithm.
Unfortunately, improving this bound further does not seem viable via this approach, since
the total number of edges contained in the graphs that belong to deeper recursive levels is
very large. Specifically, assume that we are given an n-node graph G with m edges, to-
gether with a parameter £ > 1. We can then use the j-trees in order to reduce the problem
of computing BalCutPrune on G to the problem of computing BalCutPrune on k graphs,
each of which contains roughly n/k nodes. Unfortunately, each of these graphs may have
Q(m) edges. Therefore, the total number of edges in all resulting graphs may be as large as
Q(mk), which is one of the major obstacles to obtaining faster algorithms for BalCutPrune
using j-trees.

We now provide a more detailed description of the new recursive strategy that we use in
order to prove Theorem [8.1.3]

New Recursive Strategy. We partition the vertices of the input n-vertex graph G into
k subsets Vi, Vs, ..., Vi of roughly equal cardinality, for a large enough parameter k (for
example, k = n°1)). The algorithm consists of two stages. In the first stage, we attempt to
construct k expander graphs Wy,..., Wy, where V(W;) = V; for all 1 < i < k, and embed
them into the graph G simultaneously. If we fail to do so, then we will compute a sparse
balanced cut in G. In order to do so, we run k cut-matching games in parallel. Specifically,
we start with every graph W; containing the set V; of vertices and no edges, and then perform
O(logn) iterations. In every iteration, we run the CUTORCERTIFY algorithm on each graph
Wi,..., W, in parallel. Assume that for all 1 < ¢ < k, the algorithm returns a sparse
balanced cut (A;, B;) in W;. We then use an algorithm of the matching player, that either
computes, for each 1 < i < k, a matching M, between vertices of A; and B;, and computes
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a low-congestion embedding of all matchings M;, ..., M} into graph G simultaneously, or it
returns a sparse balanced cut in GG. In the former case, we augment each graph W; by adding
the set M; of edges to it. In the latter case, we terminate the algorithm and return the sparse
balanced cut in graph G as the algorithm’s output. If the algorithm never terminates with
a sparse balanced cut, then we are guaranteed that, after O(logn) iterations, the graphs
Wi, ..., W), are all expanders (more precisely, each of these graphs contains a large enough
expander, but we ignore this technicality in this informal overview), and moreover, we obtain
a low-congestion embedding of the disjoint union of these graphs into G. Note that, in order
to execute this stage, we recursively apply algorithm CUTORCERTIFY to k graphs, whose
sizes are significantly smaller than the size of the graph G.

In the second stage, we attempt to construct a single expander graph W* on the set
{v1,..., v} of vertices, where for each 1 < ¢ < k, we view vertex v; as representing the
set V; of vertices of G. We also attempt to embed the graph W* into GG, where every edge
e = (v;,v;) is embedded into Q(n/k) paths connecting vertices of V; to vertices of V;. In
order to do so, we start with the graph W* containing the set {vy,...,v;} of vertices and
no edges and then iterate. In every iteration, we run algorithm CUTORCERTIFY on the
current graph W*, obtaining a partition (A, B) of its vertices. We then use an algorithm of
the matching player in order to compute a matching M between vertices of A and vertices
of B, and to embed every edge (v;,v;) € M of the matching into Q(n/k) paths connecting
vertices of V; to vertices of V; in graph G, with low congestion. If we do not succeed in
computing the matching and the embedding, then the algorithm of the matching player
returns a sparse balanced cut in graph GG. We then terminate the algorithm and return this
cut as the algorithm’s output. Otherwise, we add the edges of M to graph W* and continue
to the next iteration. The algorithm terminates once graph W* is an expander, which must
happen after O(logn) iterations.

Lastly, we compose the expanders W7y,..., W, and W* in order to obtain an expander
graph W that embeds into G with low congestion; the embedding is obtained by combining
the embeddings of the graphs Wy, ..., W} and the embedding of graph W*. This serves as
a certificate that G is an expander graph.

Note that the algorithm for the matching player that we need to use differs from the
standard one in that it needs to compute k different matchings between k different pre-
specified pairs of vertex subsets. Specifically, the algorithm for the matching player is given
k pairs (A1, By),...,(Ag, Br) of subsets of vertices of G of equal cardinality. Ideally, we
would like the algorithm to either (i) compute, for all 1 < i < k, a perfect matching M;
between vertices of A; and vertices of B;, and embed all edges of M; U --- U M}, into G
simultaneously with low congestion; or (ii) compute a sparse balanced cut in G. In fact
our algorithm for the matching player achieves a somewhat weaker objective: namely, the
matchings M; are not necessarily perfect matchings, but they are sufficiently large. In order
to overcome this difficulty, we introduce “fake” edges that augment each matching M; to a
perfect matching. As a result, if the algorithm fails to compute a sparse balanced cut in
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G, then we are only guaranteed that G U F' is an expander, where F' is (a relatively small)
set of fake edges. We then use a known “expander trimming” algorithm of [94] in order to
find a large subset S C V(G) of vertices, such that G[S] is an expander, and the cut S is
sufficiently sparse. We note that the notion of fake edges was used before in the context of
the cut-matching game, e.g. in [59].

The algorithm of the matching player builds on the idea of Chuzhoy and Khanna [25] of
computing maximal sets of short edge-disjoint paths, which can be implemented efficiently
via Even-Shiloach’s algorithm for decremental single-source shortest paths [36]. Unfortu-
nately, this approach requires slightly slower running time of O (m1+0(1/ " . (logm)°t?) / ¢2> ,
introducing a quadratic dependence on 1/¢, where ¢ is the conductance parameter. The ex-
pander trimming algorithm of [94] that is exploited by the cut player also unfortunately
introduces a linear dependence on 1/¢. As a result, we obtain an algorithm for the Bal-
CutPrune problem that is sufficiently fast in the high-conductance regime, that is, where
¢ = 1/polylogn, but is too slow for the setting where the parameter ¢ is low. Luckily, the
high-conductance regime is sufficient for many of our applications, and in particular it allows
us to obtain efficient approximation algorithms for maximum flow. This algorithm can then
in turn be used in order to implement the matching player, even in the low-conductance
regime, removing the dependence of the algorithm’s running time on ¢. Additional difficulty
for the low-conductance regime is that we can no longer afford to use the expander trimming
algorithm of [94]. Instead, we provide an efficient deterministic bi-criteria approximation
algorithm for the most-balanced sparsest cut problem, and use this algorithm in order to

solve the BalCutPrune problem in the low-conductance regime. This part closely follows ideas
of 20, 25], 87, 104].

8.1.2 Chapter Organization

We start with additional preliminaries in Section

For expander decomposition in the standard, unweighted case, we define the problem
to be solved by the new matching player in Section [8.3] and then provide an algorithm for
solving it. We also provide a faster algorithm the case where k = 1 (that is, the problem of
the standard matching player), which we exploit later. We prove our main technical result,
Theorem [8.1.3], in Section obtaining the algorithm for the cut player. We then prove
Theorem in Section [8.5] and then conclude with Theorem in Section [8.6]

For the weighted, custom demand setting,
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8.2 Additional Preliminaries

8.2.1 Explicit Construction of Expanders

Throughout the algorithm, we will need explicit constructions of constant-degree expanders
on any number of vertices. We state and prove the quick lemma below.

Lemma 8.2.1: Explicit expanders

Given integer n, we can construct in linear time an ag-expander X for some constant
ap > 0, such that every vertex in X has degree at most 9.

Proof. We assume that n > 10, as otherwise the graph H, with the required properties can
be constructed in constant time. We use the expander construction of Margulis [79] and
Gabber and Galil [38]. For an integer k£ > 1, let H;, be a graph whose vertex set is set
Zy X Zy, where Zy, = Z/kZ. Each vertex (x,y) € Zy x Zj has exactly eight adjacent edges,
connecting it to the vertices (z £ 2y,y), (x £ 2y + 1),y), (z,y £ 2x), and (x,y £ (2z + 1)).
Gabber and Galil [38] showed that U(H,,) = Q(1).

Given a parameter n > 10, we let k be the unique integer with (k—1)? < n < k?, and let
n’ =n— (k—1)2 Clearly, n’ < k* — (k —1)*> < 2k < (k — 1)%. In order to obtain the graph
H,, we start with the graph H;_;)2, whose vertex set we denote by V', and then add a set
V" of n' isolated vertices to this graph. Lastly, we add an arbitrary matching, connecting
every vertex of V” to a distinct vertex of V', obtaining the final graph H,. It is immediate
to verify that |V (H,)| = n, that every vertex in H has degree at most 9, and that H,, is an
(1)-expander. O

8.2.2 The Cut-Matching Game

The cut-matching game was introduced by Khandekar, Rao, and Vazirani [59] as part of
their fast randomized algorithm for the Sparsest Cut and Balanced Cut problems. We
use a variation of this game, due to Khandekar et al. [58], that we slightly modify to fit
our framework. The game involves two players - the cut player, who wants to construct an
expander fast, and the matching player, who wants to delay the construction of the expander.
Initially, the game starts with a graph H that contains an even number n of vertices and no
edges. The game is played in iterations, where in every iteration ¢, some set M; of edges is
added to the current graph H. The ith iteration is played as follows. The cut player computes
a partition (A;, B;) of V(H) with |4;|, |B;| > n/4 and |Ex(A;, B;)| < n/100. Assume without
loss of generality that |A;| < |B;|. The matching player computes any partition (A%, B}) of
V(H) with |A}| = |Bj|, such that A; C A}, and then computes an arbitrary perfect matching
M; between A} and Bj]. The edges of M; are then added to the graph H. The algorithm
terminates when graph H no longer contains a partition (A, B) of V(H) with |A|, |B| > n/4
and |Eg(A, B)| < n/100. Intuitively, once the algorithm terminates, it is easy to see that
H contains a large subgraph that is an expander. Alternatively, it is easy to turn H into
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an expander by adding one last set of O(n) edges to it. We note that the graph H is a
multi-graph, that is, it may contain parallel edges. The following theorem follows from the
result of [58] (since we slightly modify their setting, we include the proof in Appendix for
completeness).

Theorem &.2.2

There is a constant cqyq, such that the algorithm described above terminates after at
most coug logn iterations.

We will use this cut-matching game together with algorithm CUTORCERTIFY from The-
orem [8.1.3] that will be used in order to implement the cut player. The matching player
will be implemented by a different algorithm, that we discuss in the following section. Note
that, as long as the algorithm from Theorem m produces a cut (A, B) of H with the re-
quired properties, we can use the output of this algorithm as the response of the cut player.
Theorem m guarantees that, after at most O(logn) iterations of the game, the algorithm
from Theorem will return a subset S C V(H) of at least n/2 vertices, such that graph
HJ[S] is an expander. Once this happens, we will terminate the cut-matching game.

8.2.3 Expander Pruning

We use the following theorem from [94].

Theorem 8.2.3: Restatement of Theorem 1.3 from [94]

There is a deterministic algorithm, that, given a graph G = (V, E) of conductance
O(G) = ¢, for some 0 < ¢ < 1, and a collection E' C E of k < ¢|E|/10 edges of G,
computes a subgraph G’ C G \ £, that has conductance ®(G’) > ¢/6. Moreover, if
we denote A = V(G') and B = V(G)\ A, then |Eg(A, B)| < 4k, and volg(B) < 8k/¢.
The total running time of the algorithm is O(|E|/@).

We note that [94] provide a significantly stronger result, where the edges of E’ arrive in
an online fashion and the graph G’ is maintained after each edge arrival. Additionally, the
running time of the algorithm is O(k/¢?) if the algorithm is given an access to the adjacency
list of G. However, the weaker statement above is cleaner and it is sufficient for our purposes.

8.2.4 Embeddings of Graphs and Expansion
Next, we define embeddings of graphs, that will be later used to certify graph expansion.
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Definition 8.2.4: Graph embedding

Let G, H be two graphs with V(G) = V(H). An embedding of H into G is a collection
P ={P(e) | e€ E(H)} of paths in G, such that for each edge e € E(H), path P(e)
connects the endpoints of e in G. We say that the embedding causes congestion n iff
every edge ¢ € E(G) participates in at most 1 paths in P.

Next we show that, if G and H are any two graphs with |V(G)| = |V(H)|, and H is
a Y-expander that embeds into G with a small congestion, then G is also an expander, for
an appropriately chosen expansion parameter. We note that this observation was used in a

number of previous algorithms in order to certify that a given graph is an expander; see, e.g.

I10, [T, (58, 59, 66, 95].

Let G, H be two graphs with V(G) = V(H), such that H is a ¢-expander, for some
0 < ¢ < 1. Assume that there exists an embedding P = {P(e) | e € E(H)} of H into
G with congestion at most 7, for some 7 > 1. Then G is a ¢'-expander, for ¢/ = ¢ /n.

Proof. Consider any partition (A4, B) of V(G), and assume that |A| < |B|. Consider the
corresponding cut (A, B) in H, and let E' = Ey(A, B). Since H is a ¢-expander, |E'| > 1| A|.
Note that for every edge e € E’, its corresponding path P(e) in G must contain an edge

of Eg(A, B). Since the paths in P cause congestion at most 1, we get that |Eg(A, B)| >
|Eu(AB) M ]
=

8.2.5 Embeddings with Fake Edges and Expansion

In general, when using the cut-matching game, one can usually either embed an expander
into a given graph G, or compute a sparse cut S in G. Unfortunately, it is possible that |S|
is quite small in the latter case. Since each execution of the cut-matching game algorithm
takes at least Q(|E(G)|) time, we cannot afford to iteratively remove such small sparse cuts
from G, if our goal is to either embed a large expander or to compute a balanced sparse cut
in G in almost-linear time. In order to overcome this difficulty, we use fake edges (that were
also used in [59]), together with the expander pruning algorithm from Theorem [8.2.3]

Specifically, suppose we are given any graph G = (V, E), and let I’ be a collection of
edges whose endpoints lie in V', but the edges of F' do not necessarily belong to G. We
denote by G' + F' the graph obtained by adding the edges of F' to G. If an edge e lies both
in £/ and F, then we add a new parallel copy of this edge. We note that F' is allowed to be
a multi-set, in which case multiple parallel copies of an edge may be added to G.

We show that, if H is an expander graph, and we embed it into a graph G + F with a
small collection F' of fake edges, then we can efficiently compute a large subgraph of G that
is an expander.
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Lemma &8.2.6

Let G be an n-vertex graph, and let H be another graph with V(H) = V(G), with
maximum vertex degree Ay, such that H is a i-expander, for some 0 < ) < 1. Let F
be any set of k fake edges for G, and let Ag be the maximum vertex degree in G + F'.
Assume that there exists an embedding P = {P(e) | e € E(H)} of H into G+ F, that

causes congestion at most 7, for some n > 1. Assume further that k£ < 32127;77. Then

there is a subgraph G’ C G that is a ’-expander, for ¢' > & Aﬁwn’ such that, if we

denote by A = V(G') and B = V(G) \ A, then |A| > n — % and |Eg(A, B)| < 4k.

Moreover, there is a deterministic algorithm, that we call EXTRACTEXPANDER, that,
given G, H,P and F', computes such a graph G’ in time O(|E(G)|Ag - n/).

Proof. For convenience, we denote G = G+F. From Lemma graph Gisa zﬂ—expander,
for ) = ¢ /n. Moreover,
WG v

o(G) > .
(@) 2 Aqg ~ Ag-n

In the remainder of the proof, we apply Theorem m to graph G and the set F' of

edges. Recall that the set F' of fake edges has cardinality k£ < =3 A - <z 1(() ) < [EC )1|(';I>(G).

Therefore, we can use Theorem to obtain a subgraph G’ C (G \ F) C @, that has
conductance at least ( ) > Dy Gn Denotlng A=V(G) and B =V(G)\V(Q) = V(G)\
V(G), Theoremmguarantees that |Eq(A, B)| < |E4(A, B)| < 4k. Since ¥(G') > &(G),

we have that graph G' is a ¢'-expander for ¢’ = ; Aw —- The running time of the algorithm is

O(|E(G)]/®(G)) = O(|E(G)|Agn/v). Tt remains to show that |A] is sufficiently large.

Recall that Theorem [8.2.3| guarantees that |Es(A, B)| < 4k, while vol(B) < % <

%ﬁl. In particular, |B| < %f’z < 5, since k < vn_ - Since graph Gisa ﬁ—expander, and

32A¢
|Es(A, B)| < 4k, we conclude that |B| < %

| 4k akn 4kn
<3S 55 and so [A] = n — =L O

8.2.6 j-trees

Finally, for the weighted, custom demand setting, we require the concept of j-trees as defined
by Madry [78].
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Lemma 8.2.7: [7§]

There is a deterministic algorithm that, given an edge-weighted graph G = (V, E, w)

with |E| = m and capacity ratio U = Z2<c£2c together with a parameter ¢ > 1,
ecE We

computes, in time O(tm), a distribution {\;}._, over a collection of ¢ edge-weighted
graphs G, ..., Gy, where for each 1 <i <t, G; = (V, E;, w;), and the following hold:
e forall 1 <i<t, graph G, is an (M)—tree, whose core contains at most
m edges;
e for all 1 <i <t, G embeds into GG; with congestion 1; and
e the graph that’s the average of these graphs over the distribution, G = > NGy
can be embedded into G with congestion O(logm (loglogm)°W).
Moreover, the capacity ratio of each G is at most O(mU).

In particular, Definition and lemma imply that, for any cut (S, V'\ S), we have
that w(Eg,(S,V\S)) > w(Eg(S,V'\S)) for all i, and there exists i where w(Eg,(S,V\5S5)) <
B-w(Eq(S,V\S)). This is the fact that we will use in the weighted, custom demand setting.

8.3 Route or Cut: Algorithm for the Matching Player

The goal of this section is to design an algorithm that will be used by the matching player.
We use the following definition for routing pairs of vertex subsets.

Definition 8.3.1: Partial routing

Assume that we are given a graph G = (V,E), and disjoint subsets
Ay, By, Ag, Bo, ..., Ag, By of its vertices, that we refer to as terminals. Assume further
that for each 1 <i <k, |A;| < |B;|; we denote |A;| = n;. A partial routing of the sets
Ay, By, ..., A, By consists of:
e Aset M = Ule M; CV x V of pairs of vertices, where for each 1 <i < k, M;
is a matching between vertices of A; and vertices of B; (we emphasize that the
pairs (u,v) € M; do not necessarily correspond to edges of GG); and

e For every pair (u,v) € M of vertices, a path P(u,v) connecting u to v in G.

We denote the resulting routing by P = {P(u,v) | (u,v) € M} (note that the matching
M is implicitly defined by P). We say that the routing P causes congestion 7, if every
edge in G belongs to at most 1 paths in P. The value of the routing is Zle | M;|.

\. .

We are now ready to state the main result of this section, which is an algorithm that will
be used by the Matching Player. We note that the theorem is a generalization of a similar
result that was proved in [25], for the special case where k = 1.
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Theorem 8.3.2

There is a deterministic algorithm, that, given an n-vertex graph G = (V| E) with
maximum vertex degree A, disjoint subsets A;, By, ..., Ay, B, of its vertices, where
for all 1 < i < k, |A;] < |B;| and |A;| = n;, and integers z > 0, £ > 32Alogn,
computes one of the following:

* cither a partial routing of the sets Ay, By, ..., Ay, By, of value at least ) . n; — 2,

that causes congestion at most £2; or

e acut (X,Y)in G, with | X|,|Y]| > 2/2, and ¥5(X,Y) < 72Alogn/¢.

The running time of the algorithm is O(£3k|E(G)| + ¢2kn).

(We note that the parameter ¢ in the above theorem bounds the lengths of the paths in
P, that is, we will ensure that every path in P contains at most ¢ edges; however, since our
algorithm does not rely on this fact, this is immaterial).
Proof. The proof of the theorem immediately follows from the following lemma.

Lemma &8.3.3

There is a deterministic algorithm, that, given an n-vertex graph G = (V, E) with
maximum vertex degree A, disjoint subsets A, By, ..., A}, By, of its vertices, where
for all 1 <i <k, |A}| < |Bl|, and |A}| = n}, and an integer ¢ > 32Alogn, computes
one of the following:

e cither a partial routing of the sets A}, BY,..., A}, B}, in G, of value at least
(Zle né) - 818" and congestion 1; or

e acut (X,Y)in G, with [X|,[V] > (zle n) /2, and Ug(X,Y) < 72Alogn /L.
The running time of the algorithm is O(k{|E(G)| + kn).

Before we prove the lemma, we complete the proof of Theorem [8.3.2] using it. Throughout
the algorithm, we maintain the matchings My, ..., My, where M, is a matching between
vertices of A; and vertices of B;, and a routing P = {P(u,v) | (u,v) € |J, M;}. Initially, we
set M; = () for all 7, and P = (). We then iterate. In every iteration, for each 1 < i < k, we
let A; C A; and B, C B; be the subsets of vertices that do not participate in the matching
M;, and we denote n); = | Al|; since |A;| < |B;|, we are guaranteed that |A;| < |Bi|. We also
denote N' = > ".n.. If N’ < z, then we terminate the algorithm, and return the current
matchings My, ..., My, together with their routing P. Otherwise, we apply Lemma [8.3.3
to graph G and vertex sets A}, By,..., A}, B). If the outcome is a cut (X,Y) in G, with
| X|,|Y] > N'/2, and ¥¢(X,Y) < 72Alogn/¢, then we terminate the algorithm, and return
the cut (X,Y’). Notice that, since N’ > z holds, we are guaranteed that |X]|, |Y| > z/2,
as required. Therefore, we assume from now on that, whenever Lemma [8.3.3] is called, it
returns a partial routing ((Mj,..., M), P’') of the vertex sets A}, B],..., A}, By, of value
at least SN;#, that causes congestion 1. We then add the paths in P’ to P, and for each
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1 <i <k, we add the matching M/ to M;, and continue to the next iteration.
The key in the analysis of the algorithm is to bound the number of iterations. For
all j > 1, let NI denote the parameter N’ at the beginning of iteration j. Then, since

Lemma|8.3.3|returns a routing of value at least %, we get that N7, < N;(1—-8logn/¢?).
Therefore, after ¢? iterations, parameter N 7 is guaranteed to fall below z, and the algorithm
will terminate. Notice that the congestion of the final routing P is bounded by the number
of iterations, £2. Moreover, since the running time of each iteration is O(kl|E(G)|+ kn), the
total running time of the algorithm is O(k¢*|E(G)| + knf?). In order to complete the proof
of Theorem [8.3.2] it is now enough to prove Lemma [8.3.3]

Proof of Lemma Our algorithm is very similar to that employed in [25], and consists
of two phases. In the first phase, we employ a simple greedy algorithm that attempts to
compute a partial routing of sets A, By, ..., A}, B;. If the resulting routing contains enough
paths then we terminate the algorithm and return this routing. Otherwise, we proceed to
the second phase, where we compute the desired cut.

Phase 1: Route. We use a simple greedy algorithm. Initially, we set, for all 1 < i < k,
M; = (), and we set P = (). The algorithm then iterates, as long as there is a path P in G
of length at most ¢, that, for some 1 < i < k, connects some vertex v € A to some vertex
u € Bl. The algorithm computes any such path P, adds (u,v) to M;, and adds the path
P to P, denoting P = P(u,v). We then delete every edge of P from G, and we delete u
from A} and v from Bj, and then continue to the next iteration. The algorithm terminates
when, for each 1 < ¢ < k, every path in the remaining graph G connecting a vertex of A}
to a vertex of B] has length greater than ¢ (or A, = )). It is easy to verify that, for each
1 <i <k, the final set M; is a matching between vertices of A} and vertices of BY, and that
P is a collection of edge-disjoint paths, of length at most ¢ each, containing, for every pair

(u,v) € U, M;, a path P(u,v) connecting v to v in G. If >, |M;| > (Zle né) Slogn ' then
we terminate the algorithm, obtaining the desired partial routing. Otherwise, we continue
to the second phase, where a cut (X,Y) will be computed.

We implement the algorithm for the first phase by using Even-Shiloach trees.

Lemma 8.3.4: [32, [36]

There is a deterministic data structure, called ES-tree, that, given an unweighted
undirected n-vertex graph G undergoing edge deletions, a root node s, and a depth
parameter ¢, maintains, for every vertex v € V(G) a value 0(s,v) such that (s,v) =
distg (s, v) if distg(s,v) < £ and §(s,v) = oo otherwise (here, distg (s, v) is the distance
between s and v in the current graph G). The data structure supports shortest-
paths queries: given a vertex v, return a shortest path connecting s to v in G, if
distg(s,v) < £, and return oo otherwise. The total update time of the data structure
is O(|E(G)|¢ 4+ n), and time needed to process each query is O(|P]), where P is the
path returned in response to the query.
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We construct k graphs G,..., Gk, where graph G; is obtained from a copy of G, by
adding a source vertex s; that connects to every vertex in A, with an edge, and a destination
vertex t;, that connects to every vertex in B with an edge. For each 1 < i < k, we then
maintain an ES-tree in graph G;, from source s;, up to depth /42. Note that the total update
time needed in order to maintain all these ES-trees under edge deletions is O(¢k|E(G)|+kn).
Our algorithm processes the graphs GG; one-by-one. When graph G} is processed, we perform
a number of iterations, as long as distg,(s;,t;) < ¢+ 2. In each such iteration, we perform a
shortest-path query in the corresponding ES-tree for vertex t;, obtaining a path P, of length
at most £ + 2, connecting s; to t;. By discarding the first and the last edge on this path, we
obtain a path P’ of length at most ¢, connecting some vertex v € A to some vertex u € B].
We delete all edges on path P’ from all copies Gy, ..., G}, of the graph G, and we delete v
and v from G}, updating all corresponding ES-trees. Note that the total time to respond to
all queries is O(|E(G)]), as whenever a path P is returned, all its edges are deleted from all
graphs G;. Therefore, the total running time of the algorithm is O(k(|E(G)| + kn).

Phase 2: Cut. We use the following standard algorithm that follows the ball-growing
paradigm.

Claim 8.3.5

There is a deterministic algorithm, that, given an unweighted n’-vertex graph H’ with
maximum vertex degree at most A, and two sets S, T of its vertices, such that every
path connecting a vertex of S to a vertex of T" in H’ has length greater than ¢, for
some parameter ¢ > 1 computes, in time O(|E(H')|), a cut Z in H’', such that:

* |Z] <n'/2;

e cither S C Z or T' C Z hold; and

° |Ew(Z,V(H)\ 2)| < 2252 . | 7.

. J

Proof. Let Sy = S, and for all j > 0, let S; contain all vertices of S;_;, and all neighbors
of vertices of S;_; in graph H'. We also define T, = T, and for all j > 0, we let 7} contain
all vertices of T;_;, and all neighbors of vertices of Tj_; in graph H’. We need the following
standard observation:

Observation 8.3.6

There is an index 0 < j < [£/4], such that either (i) |S;11]| < n'/2 and |Eg/(S;, V(H')\

Sy)| < B -|S]; or (i) [Tja| < //2 and |Epi (T3, V(H') \ S;)] < 2985 |T).

Proof. Assume for contradiction that the claim is false. Let 7' be the smallest index, such
that |Sy| > n'/2 or |Tj| > n’/2. Assume w.l.o.g. that |Tj| > n'/2.

Assume first that j* < ¢/2. Then for all 1 < j < [£/4], |S;| < n//2 must hold (as
otherwise, there is a path connecting a vertex of S to a vertex of T', of length at most /).
However, from our assumption, for all 0 < j < [£/4], |Eg/(S;, V(H') \ S;)| > SAIT?’C’T”, - 195
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Since the maximum vertex degree in H' is bounded by A, we get that |Sj11\.S;| > Sloég n |S;],

/ N [€/4] /
and so |Sj41| > |9)] (1  Blogn ) Overall, we get that |Spga| > [So| - (1 + Sloan > > %,

7
a contradiction.

Assume now that j° > ¢/2. Then we get that for all 1 < j < [¢/4], |T;| < n/2 must
hold. Applying the same reasoning as above to sets Tj, we conclude that |17,4| > n//2, a
contradiction. 0

The algorithm performs two BFS searches in H’ simultaneously, one starting from S and
another starting from 7', until an index j with the properties guaranteed by Observation[8.3.6]
is found. If |S;41] < n'/2 and |Eg/(S;, V(H')\ ;)| < %ﬁjg”/ - |S;], then we return Z = S;;
otherwise, and otherwise we return Z = Tj. O

We are now ready to describe the algorithm for Phase 2. For convenience, we denote
N = Zle n;. Recall that Phase 2 is only executed if the routing P computed in Phase 1
contains fewer than 8]\7;# paths. Let E’ be the set of all edges lying on the paths in P, so
|E'| < B8 (as the length of every path in P is at most £), and let H = G\ E’. We also
denote, for all 1 < i < k, by A7 C A’ the subset of all vertices of the original set A, that
do not participate in the matching M;, and we define B/ C B; similarly. Notice that for all
1<i <k, if AY, B! # (), then the length of the shortest path, connecting a vertex of A? to
a vertex of B! is greater than /.

Our algorithm is iterative. We maintain a subgraph H' of H, that is initially set to
be H. In every iteration ¢, we compute a subset U; C V(H') of vertices of H’', such that
\Ui| < |V(H")|/2, and |Ep:/(U;, V(H') \ U;)| < SAI% -|Ui]. We then delete, from graph H’,
all vertices of U;, and continue to the next iteration. Throughout the algorithm, we may
update the sets A7 and B}, by removing some vertices from them.

The algorithm is executed as long as there is some index 1 < j < k, with A7, BY # 0,
and as long as ||J, U;| < n/4; if either of these conditions do not hold, the algorithm is
terminated. We now describe the ith iteration of the algorithm, and we let 1 < j < k be an
index for which A7, B # (). We apply the algorithm from Claim m to the current graph
H', and the sets S = A7, T = BY of vertices; recall that every path connecting a vertex of
A% to a vertex of BY in H' has length greater than (. Let Z be the cut returned by the
algorithm. We set U; = Z. We also denote by E; = Ey/(Z,V(H') \ Z). Recall that we are
guaranteed that |E;| < 2182 . |U;|. Moreover, either AY C U, or BY C U;. We update the
current graph H’, by deleting the vertices of U; from it. For all 1 < j’ < k, we delete from

;-’, and from B;-’, all vertices that lie in the set U;.

Let g be the number of iterations in the algorithm; it is easy to see that ¢ < k. Therefore,
the running time of the algorithm in Phase 2 so far is O(k - |[E(H)|) = O(k - |[E(G)]). Let
U=U_,U;,andlet E=J_, E.

If the algorithm terminated because |U| > n/4, then we are guaranteed that |U| > N/2,
as N < n/2 must hold. Otherwise, we are guaranteed that for all 1 < j < k, either A;’ =10
(and so A5 C U), or Bf =0 (and so B} C U). In the latter case, we get that:
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8N logn > N/2.

k
U] >> nf—|P|>N - a2

J=1

since we have assumed that ¢ > 32Alogn. Moreover, it is immediate to verify that
|| < S29En U,

Consider now the original graph H. We define a cut (X,Y’) in H by setting X = U and
Y = V(H)\U. Since |E(G)\ B(H)| = |E'| < %50 < Lltlgn e get that |Eq(X,Y)| <
[B]+|E| < 285 1],

Next, we claim that |X| < 3n/4. Indeed, we are guaranteed that Y% |U;| < n/4, and

so U, < w We then get that altogether, | X| = Y"1, |U;| < § + w <3 In
particular, |Y| > n/4 and so |Y| > | X|/3. Therefore, |Eg(X,Y)| < 28080 . | x| < 2ajsn
min {|X|, |Y|}, and so ¥g(X,Y) < m#‘ As observed already, |X| > N/2 = Y. n}/2,

[l

and |Y|>n/4>>".n;/2, as ) . n; < n/2 must hold.

The following corollary follows immediately from Theorem by setting the parameter
¢ =144Alogn /1.

Corollary 8.3.7

There is a deterministic algorithm, that we call ROUTEORCUT, that, given an n-vertex

graph G = (V, E) with maximum vertex degree A, disjoint subsets Ay, By, ..., Ay, By

of its vertices, where for all 1 <1i <k, |4;| < |B;| and |A4;| = n;, an integer z > 0, and
a parameter 0 < ¢ < 1/2, computes one of the following:

* cither a partial routing of the sets Ay, By,. .., Ay, By, of value at least ). n; — z,
that causes congestion at most O(A?log®n/1?); or

e acut (X,Y)in G, with | X|,|Y] > 2/2, and ¥5(X,Y) <.
The running time of the algorithm is O(A3k|E(G)|/4? + kA2n/1)?).

An Improved Algorithm for £ =1

For the special case where k = 1, we provide a somewhat faster algorithm, summarized in
the following theorem. We note that this algorithm is not essential for the proof of our main
result (Theorem , but we can use it to provide a self-contained proof of the theorem
with a somewhat slower running time, which we believe is of independent interest.
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Theorem 8.3.8

There is a deterministic algorithm, that we call ROUTEORCUT-1PAIR, that, given a
connected n-vertex m-edge graph G = (V, F) with maximum vertex degree A, two
disjoint subsets Ay, By of its vertices, where |A;| < |B;| and |A;| = ny, an integer
z > 0, and a parameter 0 < ¢ < 1/2, computes one of the following:
e cither a partial routing of the sets Ay, By, of value at least n; — z, that causes
congestion at most 4A /1; or

e acut (X,Y)in G, with | X|[,|Y]| > z/A, and Vg(X,Y) < 9.
The running time of the algorithm is O (”ml%)

Proof. Theorem [8.3.8]is an easy application of either the bounded-height variant of the push-
relabel-based algorithm of Henzinger, Rao and Wang [51] for max-flow, or the bounded-height
variant of the blocking-flow-based algorithms by Orrecchia and Zhu [89] [

We start by introducing some basic notation. Suppose we are given an unweighted
undirected graph G = (V, E). Welet S : V' — Z>( denote a source function and T : V' — Z>q
denote a sink function. For a vertex v € V, we sometimes call T'(v) its sink capacity.
Intuitively, initially, for every vertex v € V, we have S(v) units of mass (substance that
needs to be routed) placed on vertex v. Additionally, every vertex v € V may absorb up to
T'(v) units of mass. Our goal is to route the initial mass across the graph (using standard
single-commodity flow) so that all mass is absorbed. We use a flow function f : V xV — R,
that must satisfy: (i) for all u,v € V, f(u,v) = —f(v,u); and (ii) if (u,v) ¢ E, then
f(u,v) = 0. Whenever f(u,v) > 0, we interpret it as f(u,v) units of mass are sent via the
edge (u,v) from u to v, while f(u,v) < 0 means that the same amount of mass is sent in the
opposite direction.

We require that Y, S(v) <>, T(u), that is, the total amount of mass that needs to
be routed is bounded by the total sink capacities of the vertices. Given a flow f: V xV — R,
the congestion of f is max.cp |f(e)|. We say that f is a preflow if, for every vertex v € V,
Y wey J(v,u) < S(v); in other words, the net amount of mass routed away from any node v
is bounded by the amount of the source mass S(v). For every vertex v € V', we also denote
by f(v) = S(v) + > ,cv f(u,v) the amount of mass that remains at v after the routing f.
We define the absorbed mass of a node v as aby(v) = min{f(v),T(v)}, and the excess of v
as exs(v) = f(v) — aby(v), measuring the amount of flow that remains at v and cannot be
absorbed by it. Note that, if ex;(v) = 0 for every vertex v, then all the mass is successfully
routed to the sinks. Let ex;(V) = ) exy(v) denote the total amount of mass that is not
absorbed by the sinks.

The following lemma easily follows from Theorem 3.3 in [88] (or Theorem 3.1 in [51]).

'Both algorithms are designed to have local running time, that is, they may not read the whole graph.
However, we do not need to use this property here.
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Lemma &.3.9

There is a deterministic algorithm, that, given an m-edge graph G = (V, E'), a source
function S : V' — Z>, a sink function 7" : V' — Z>(, and a parameter 0 < ¢ < 1,
such that > _, S(v) < >, o, T'(v), and for every vertex v € V, S(v) < degg(v) and

T(v) < degy(v), computes, in time O (@%), an integral preflow f of congestion at

most 4/¢. Moreover, if the total excess exs(V') > 0, then the algorithm also computes
a cut (9,S) with ®5(S) < ¢ and volg(S), volg(S) > ex; (V).

We are now ready to complete the proof of Theorem [8.3.8, For convenience, we denote
Ay by A, By by B, and n; by N. For the input graph G = (V| E), we define a source function
as follows: for all v € A, S(v) = 1, and for all other vertices, S(v) = 0. Similarly, we define
the sink function to be T'(v) =1 if v € B, and T'(v) = 0 otherwise.

We then apply the algorithm from Lemma to graph G, source function S, sink
function 7" and parameter ¢ = ¢/A. Let f be the resulting preflow with congestion at most

4/¢ < 4A /1. The running time of the algorithm is O (@%) =0 (%)

We now consider two cases. The first case happens when ex;(V) > z. In this case, we
obtain a cut (X,Y) with ®¢(X,Y) < ¢ and volg(X), volg(Y) > ex;(V) > 2. Since the
maximum vertex degree in GG is bounded by A, we get that |X|,|Y| > z/A. Moreover,
Ua(X,Y) <AP(X,Y) < Ap < 9.

Consider now the second case, where ex;(V) < z. Let B’ be a multi-set of vertices, where
for each vertex v € V, we add exs(v) copies of v into B’ (since f is integral, so is exs(v)
for all v € V). Then |B’| < z, and f defines a valid integral flow from A to B U B’, with
congestion at most 4A /1, such that all but at most z flow units terminate at distinct vertices
of B. It now remains to compute a decomposition of f into flow-paths, and then discard
the flow-paths that terminate at vertices of B’. This can be done by using, for example,
the link-cut tree [98|, or simply a standard Depth-First Search. For the latter, construct a
graph G’, obtained from G by creating |f(e)| parallel copies of every edge e € E(G), that
are directed along the direction of the flow f on e; recall that |f(e)] < 4A/¢. We also add
a source s that connects to every vertex of A with a directed edge. We then perform a DFS
search of the resulting graph G’, starting from s. If the DFS search leaves some vertex v
without reaching any vertex of B U B’, then we delete v from the graph G’. If the search
reaches a vertex v € B U B’ then we retrace the current path from s to v, adding it to the
path-decomposition that we are constructing, and deleting all edges on this path from G'.
We then restart the DFS search. It is easy to verify that every edge is traversed at most twice
throughout this procedure, and so the total running time is O|E(G")| = O(|E(G)| - A/v).
Let P be the final collection of paths that we obtain. Then every vertex of A has exactly one
path in P originating from it, and all but at most z paths in P terminate at distinct vertices
of B. We discard from P all paths that do not terminate at vertices of B, obtaining the

desired final collection of paths. The total running time of the algorithm is O <%ﬂ). O
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8.4 Deterministic Cut-Matching Game: Proof of Theo-
rem [8.1.9

The goal of this section is to prove Theorem We do so using the following theorem, that
can be thought of as a restatement of Theorem [8.1.3]in a way that will be more convenient
to work with in our inductive proof. Recall that cqy is the constant from Theorem [8.2.2]

Theorem 8.4.1

There are universal constants ¢y, Ny and a deterministic algorithm, that, given an
n-vertex graph G = (V| F) and parameters N, q with N > Ny an integral power of 2,
and ¢ > 1 an integer, such that n < N?, and the maximum vertex degree in G is at
most cene logn, computes one of the following:

e cither a cut (A4, B) in G with |A|,|B| > n/4 and |Eg(A, B)| < n/100; or
e asubset S C V of at least n/2 vertices, such that ¥(G[S]) > 1/ (¢log N)*.
The running time of the algorithm is O (N‘“’1 - (qlog N)CO‘I2>.

We first show that Theorem [8.1.3 follows from Theorem [8.4.1] The parameter Ny in
Theorem [8.1.3 remains the same as that in Theorem [8.4.1] Assume that we are given an
n-vertex graph and a parameter r, such that n'/7 > Ny. We set ¢ = r, and we let N be
the smallest integral power of 2 such that N > n'/% observe that (N/2)? < n < N? and
N > Ny hold. Moreover, since qlog(N/2) < logn, if Ny is a large enough constant, then
qlog N < 2logn.

We apply the algorithm from Theorem [.4.1] to graph G with the parameter ¢. If the
outcome is a cut (A, B) with |A|, |B| > n/4 and |E(A, B)| < n/100, then we return this cut as
the outcome of the algorithm. Otherwise, we obtain a subset S C V of at least n/2 vertices,
such that U(G[S]) > 1/ (glog N)* > 1/ (2logn)™ > @ (1/(logn)°™), as required. Lastly,

the running time of the algorithm is O (N‘IJrl - (qlog N)00q2> =0 (nHO(l/T) - (log n)O(TQ)).

The remainder of this section is dedicated to proving Theorem [8.4.1L The proof is by
induction on the parameter q. We start with the base case where ¢ = 1 and then show the
step for ¢ > 1.

8.4.1 Base Case: ¢ =1

The algorithm uses the following key theorem.
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Theorem 8.4.2

There is a deterministic algorithm that, given as input a graph G’ = (V', E’) with
|V'| = n’ and maximum vertex degree A = O(logn’), in time O((n’)?) returns one of
the following;:
e cither asubset S C V' of at least 2n//3 vertices such that G'[S] is an Q(1/ log® n/)-
expander; or

e acut (X,Y) in G’ with | X[,|Y] > Q(n'/log’ n’) and ¥ (X,Y) < 1/100.

We prove Theorem below, after we complete the proof of Theorem for the
case where ¢ = 1 using it. Our algorithm performs a number of iterations. We maintain
a subgraph G’ C G, at the beginning of the algorithm, G’ = G. In the ith iteration, we
compute a subset S; C V(G’) of vertices, and then update the graph G’ by deleting the
vertices of S; from it. The iterations are performed as long as ||J, S;| < n/4.

In order to execute the ith iteration, we consider the current graph G’, denoting |V (G")| =
n'. Note that, since we assume that | J, _, Si| < n/4, we get that n’ > 3n/4. We then apply
Theorem to graph G’. If the outcome is a subset S C V' of at least 2n’/3 vertices such
that G'[S] is an Q(1/ log® n’)-expander, then we terminate the algorithm and return .S; in this
case we say that the iteration terminated with an expander. Notice that, since n’ > 3n/4,
and |S| > 2n'/3, we are guaranteed that |S| > n/2. Moreover, assuming that Ny is a large
enough constant, the expansion of G[S] is at least Q(1/log’n/) > 1/log®n > 1/log® N
as required. Otherwise, we obtain a cut (X,Y) in G’ with |X|,|Y] > Q(n//log’n’) and
Ve (X,Y) < 1/100; in this case we say that the iteration terminated with a cut. Assume
w.l.o.g. that | X| <|Y|. We then set S; = X, update the graph G’ by removing the vertices
of S; from it, and continue to the next iteration. If the algorithm does not terminate with
an 1/log® N-expander, then it terminates once ||J, S| > n/4 holds. Let i denote the
number of iterations in this case. Since we are guaranteed that ||J,_, Sv| < n/4, while
S| < n//2 < 3n/8, we get that n/4 < |U,_, Su| < 5n/8. Let A = |J,_, Sy, and let
B = V(G) \ A. From the above discussion, we are guaranteed that |A|,|B| > n/4, and
moreover, since the cut Sy that we obtain in every iteration ¢’ has sparsity at most 1/100 in
its current graph G', it is easy to verify that |Eg(A, B)| < |A|/100 < n/100. We then return
the cut (A, B) as the algorithm’s outcome. Since for all 1 < i’ <, [Sy| > Q(n/log’ n), the
number of iterations is bounded by O(log® n), and so the total running time of the algorithm
is O(n?) = O(N?log® n), if ¢, is a large enough constant. In the remainder of this subsection
we focus on the proof of Theorem [8.4.2]

Proof of Theorem [8.4.2]

As our first step, we use Lemma to construct in linear time a *-expander H = H,/
on n’ vertices, with ¢* = W(H) = Q(1), such that maximum vertex degree in H is at most
9. This can be done using standard explicit constructions of expanders; see Theorem 2.4
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of [27] for a proof. We identify the vertices of H with the vertices of G’, so V(H) = V'. The
running time of this step is O(n'). Using a simple greedy algorithm, and the fact that the
maximum vertex degree in H is at most 9, we can partition the set E(H) of edges into 17
matchings, M, ..., Mi;. We then perform up to 17 iterations; in each iteration i, we will
either embed the edges of M; into G’, after possibly adding a small number of fake edges to
it, or we will compute the desired cut (A, B) in G'.

The ith iteration is executed as follows. We denote M; = {ey, ..., ek}, where the edges
are indexed in an arbitrary order. For each 1 < j < k;, denote e; = (u;,v;). We define two
corresponding sets Aj;, B; of vertices of G', where A; = {u;} and B; = {v;}. We then apply
Algorithm ROUTEORCUT from Corollary to graph G', the sets Ay, By, ..., Ay, By, of

its vertices, integer z = | —%— | for some large enough constant ¢, and parameter 1) = 1/100.
clog®n

Recall that the running time of the algorithm is O (k;| E(G")| A3 /?+k;n' /A%) = O((n')?). We
now consider two cases. If the algorithm returns a cut (X,Y), with ¥/ (X,Y) < 4, then we
terminate the algorithm and return this cut; in this case, | X1, [Y| > 2/2 > Q(n// log® n’) must
hold. Otherwise, the algorithm computes a partial routing P of the sets Ay, By, ..., A, By,
of value at least k; — z, that causes congestion at most O(A?log?n’/¢?) = O(log* n’). Let
M, C M; be the subset of edges that are routed in P, so for every edge e; € M, there is
a path P(e;) € P connecting its endpoints. Let M C M, denote the set of the remaining
edges, so |M]'| < z. We let F; = M/ be a set of fake edges in graph G’, that we use in order
to route the edges of M. For each edge e; € M/, we let P(e;) be the path consisting of the
new fake copy of e; in G'. Let P; = PU{P(e;) | e; € M!}. We now obtained an embedding
of the edges of M; into G’ + Fj, with congestion O(log” n/).

If the algorithm never terminates with the cut (X,Y") with U (X,Y) < 9, then, after 17
iterations, we obtain an embedding P* = Uj; P; of H into G + F', where F' = Uzlil F;is a
set of at most 17z fake edges; the congestion of the embedding is n = O(log® n’). Moreover, if
we denote by Ag the maximum vertex degree in G+ F', then Ag < 17+ A < 17A. Next, we
apply Algorithm EXTRACTEXPANDER from Lemma to graphs G’, H, the set F of fake

edges, and the embedding P* of H into GG. Since 3721’;’;'7] > o1 A‘blzgil ) > 0(107;/5 oy by letting
¢*n/

the constant ¢ used in the definition of z be large enough, we ensure that |F'| < 17z < 35 Ao
as required. The algorithm from Lemma then computes a subgraph G” C G’ that is a

1)’-expander, where v’ > 6Kcn = <10g+n, , with:

417z

V(G”) > n' ¢*

n' — O(zlog*n)

By letting ¢ be a large enough constant, we can ensure that \V~(G” )| > 2n'/3. The running
time of Algorithm EXTRACTEXPANDER from Lemma is O(|E(G")|Agn/v*) = O(n),
and so the total running time of the algorithm is O((n')?).
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8.4.2 Step: ¢ >1

Suppose we are given an integer ¢ > 1. We assume that Theorem holds for ¢ — 1:
that is, there is a deterministic algorithm, that we denote by A(q — 1), that, given an n-
vertex graph G with maximum vertex degree at most ceyglogn and n < N971, for some
N > Ny, either returns a cut (A, B) in G with |A|,|B| > n/4 and |E(A, B)| < n/100, or
it computes a subset S C V(@) of at least n/2 vertices, such that U(G[S]) > 1,-1, where
Vg1 =1/ ((g — 1)log N)3@=D). We denote the running time of this algorithm by T'(¢—1) =

O <Nq -((¢g—1)log N)Co(q*1)2>. Throughout the proof, we also denote ¢, = 1/ (qlog N)®)

We now prove that the theorem holds for the given value of ¢, by invoking Algorithm
A(q— 1) a number of times. The following theorem is central to proving the induction step.

Theorem &8.4.3

There is a deterministic algorithm that, given as input an n/-vertex graph G’ = (V' E’)
and integers N, ¢ with N > Ny an integral power of 2 and ¢ > 1, such that N7 !/2 <
n’ < N9 and maximum vertex degree of G’ is A = O(logn’), returns one of the
following;:

e cither a subset S C V’ of at least 2n’/3 vertices such that G'[S] is a ,-~expander;
or

e acut (X,Y)in G with |X|,[Y] > ©Q (wn) and Wer (X, Y) < 1/100.

log® n’

The running time of the algorithm is O (N4 - (glog N)¥+°M) + O (N -logn/) - T(q—
1).

We prove Theorem [8.4.3] below, after we complete the proof of Theorem for the
current value of ¢ using it.

Note that we can assume that n > N?7!| since otherwise we can use algorithm A(g — 1),
to either compute a cut (A, B) in G with |A],|B| > n/4 and |E(A, B)| < n/100, or to
compute a subset S C V(G) of at least n/2 vertices, such that U(G[S]) > ¢,—1 > 9, in
time T(q —1) = O (Nq (g —1)log N)CO(‘I*N).

Our algorithm performs a number of iterations. We maintain a subgraph G’ C G; at the
beginning of the algorithm, G’ = G. In the ith iteration, we compute a subset S; C V(G’) of
vertices, and then update the graph G’ by deleting the vertices of S; from it. The iterations
are performed as long as ||J, S;| < n/4.

In order to execute the ith iteration, we consider the current graph G, denoting |V (G')| =
n'. Note that, since we assume that || J,_, S| < n/4, we get that n’ > 3n/4, and in particular
N971/2 < n/ < N9. We then apply Theorem to graph G’. If the outcome is a subset
S C V' of at least 2n’/3 vertices such that G'[S] is a i,-expander, then we terminate the
algorithm and return S. Notice that, since n’ > 3n/4, and |S| > 2n'/3, we are guaranteed

that |S| > n/2. Otherwise, we obtain a cut (X,Y) in G' with |X|,|Y] > Q (wqu/) and

log® n’
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Ve (X,Y) <1/100. Assume w.l.o.g. that | X| < |Y|. We then set S; = X, update the graph
G’ by removing the vertices of S; from it, and continue to the next iteration. If the algorithm
does not terminate with a 1,-expander, then it terminates once || J, Sir| > n/4 holds. Let i
denote the number of iterations in this case. Since we are guaranteed that | J,_; Sv| < n/4,
while |S;] < n'/2 < 3n'/8, we get that n/4 < [J,_, Sv| < 5n/8. Let A = |J,_, Sy, and
let B =V(G)\ A. From the above discussion, we are guaranteed that |A|, |B| > n/4, and
moreover, since the cut Sy that we obtain in every iteration ¢’ has sparsity at most 1/100 in
its current graph G', it is easy to verify that |Eg(A, B)| < |A|/100 < n/100. We then return
the cut (A, B) as the algorithm’s outcome.

Notice that the number of iterations in the algorithm is bounded by:

O(log® n/1by-1) = O ((qlog N)¥@™ -1og” n) < O ((qlog N)**'),

since n < NY. Therefore, the total running time of the algorithm is at most:

O (N1 (qlog N)'*79W) + O (N(qlog N)**?) - T(q — 1).

From the induction hypothesis, T(¢ — 1) = O (Nq -((¢g—1)log N)Co(q*1)2>. Assuming
that ¢ > 1, and that ¢y is a large enough constant, we get that the running time is T(q) =
@) <N‘1+1 - (qlog N)C°q2>, as required.

In the remainder of this subsection we focus on the proof of Theorem [8.4.3]

Proof of Theorem [8.4.3

One of the main technical tools in the proof of the theorem is composition of expanders that
we discuss next.

Composing Expanders. Suppose we are given a collection {G1, ..., G} } of disjoint graphs,
where for all 1 < ¢ < h, the set V(G;) of vertices, that is denoted by V;, has cardinality at
least N. Let H be another graph, whose vertex set is {vy,...,v,}. An N-composition of H
with G, ..., Gy is another graph G, whose vertex set is U?:l V;, and whose edge set consists
of two subsets: set E' = I, E(G;), and another set E? of edges, defined as follows: for
each edge e = (v;,v;) € E(H), let M(e) be an arbitrary matching of cardinality N between
vertices of V; and vertices of Vj. Then E? = |J,cpy) M(e). The following theorem shows
that, if each of the graphs G, ..., Gy is a y-expander, and graph H is a ¢/’-expander, then
the resulting graph G is also an expander for an appropriately chosen expansion parameter.
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Theorem 8.4.4

Let Gy,...,Gy be a collection of h > 1 graphs, such that for each 1 <7 < h, N <
[V(G;)| < yN, and G is a ¢-expander, for some N > 1,v>1,and 0 < ¢ < 1. Let H
be another graph with vertex set {vy, ..., v}, such that H is a ¢/’-expander, and let A

be maximum vertex degree in H. Lastly, let G be a graph that is an N-composition
of H with G, ...,Gy. Then graph G is a ¢"-expander, for ¢" = ' /(16A~?).

Proof. For convenience, for all 1 < ¢ < h, we denote V(G;) by V;. Let (A, B) be any partition
of V(G). It is sufficient to prove that |Eg(A, B)| > ¢” - min {|A|, |B|}.

Consider any graph G;, for 1 < i < h. We say that G; is of type 1 if |V; N A| >
(1 - 2%) |Vi], and we say that it is of type 2 if |V; N B| > (1 - %) |Vi]. Notice that a graph
G, cannot belong to both types simultaneously, and it is possible that it does not belong to
either type. Let N; be the number of type-1 graphs G;, and let Ny be the number of type-2
graphs. Assume w.l.o.g. that Ny < Ny. Let S C V(H) contain all vertices v;, such that G;
is a type-1 graph, so |S| = N;. Since graph H is a ¢’-expander, |Ey (S, V(H)\ S)| > ¢'|5)].

We partition the set A of vertices into two subsets: set A’ contains all vertices that lie in
type-1 graphs G;, and set A” contains all remaining vertices. Recall that graph G contains,
for every edge e = (v;,v;) € Ex(S,V(H) \ S), a collection M(e) of N edges, connecting
vertices of V; to vertices of V;. Consider any such edge e = (v;,v;), with v; € S. Since
VinA| > <1 - %) \Vil, and |V;| < yN, |V;NB| < ‘2%' < & Therefore, at least N/2 edges of
M (e) have one endpoint in A’; the other endpoint of each such edge must lie in A” U B. We
conclude that |Eg(A", A” U B)| > N'|EH(S’2V(H)\S)‘ > 1/;’]\2/|S|' Since every graph G; contains
between N and yN vertices, we get that |A’| < yN|S|, and so |Eg(A", A" U B)| > %.
Since maximum vertex degree in H is A, every vertex in A” may be an endpoint of at most
A such edges.

We now consider two cases. First, if |[A”| < ¢'|A'|/(4A7), then |Eq(A’, A”)| < AJA”| <
V'|A’/(47). Therefore, |[Ec(A, B)| 2 |Eq(A’, B)| > /| A'|/(4y) = ¢'|A[/(8y) = ¢"|A].

Lastly, assume that |A”| > ¢/|A’|/(4A%), so |A”| > /| A|/(8A7). Consider any graph
G; that is not a type-1 graph, so |[V; N A| < (1 — %) \Vi|. If |V 0 Al < |V;|/2, then there
are at least ©|V; N A| edges of G; in Eg(A, B). Otherwise, there are at least ¢|V; N B]
edges of G; in Eg(A, B). Since |V; N B| > |Vi|/2y > |Vi N A|/(27), the number of edges
that G; contributes to Eg(A, B) is at least ¥|V; N B| > 9¥|V; N A|/y. We conclude that
|Ec(A, B)| > ¥|A"|/(27) = v/ |A]/(16A5°) > "] Al. O

Proof Overview. We now provide an overview of the proof of Theorem [8.4.3] and set up
some notation.
In order to simplify the notation, we denote the input graph by G = (V, E), and we denote

V| =n and |E| = m; recall that |E| = O(nlogn). Let N' = N'/2, and let N = Ln/N’J,

so N < 2N. Since N is an integral power of 2, N’ is an even integer. Moreover, from our
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assumption that n > N971 /2 we get that N > 1.

We partition the set V of vertices into N + 1 subsets Vi,.., Vi, Vi, where sets
Vi, ..., Vg have cardinality exactly N’ each, and the last set, that we denote by Z = Vg,
has cardinality less than N’. We call the vertices in Z the extra vertices.

The algorithm consists of three steps. In the first step, we construct expanders Hy, ..., Hg,
where for all 1 <i < N, V(H;) = V;, that we attempt to embed into G. We will either suc-
ceed in embedding these expanders with a small congestion and a relatively small number of
fake edges, or we will compute the desired cut (X,Y’) in G. In the second step, we construct
an expander H' whose vertex set is vy,..., vy, where we think of vertex v; as representing
the set V; of vertices of G. We will attempt to embed graph H’ into G, with a small number
of fake edges and low congestion, where every edge e = (v;,v;) of H' is embedded into N’
paths connecting vertices of V; to vertices of V; in G. If our algorithm fails to find such an
embedding, then we will again produce the desired cut (X,Y’) in G. If, over the course of the
first two steps, the algorithm does not terminate with a cut (X,Y) in GG, then we consider an
expander H*, obtained by computing a N’-composition of Hy, ..., Hy and of H’, and then
adding the vertices of Z, together with a matching connecting every vertex of Z to some
vertex of V3 U---U Vg to the resulting graph. The algorithm from the first two steps has
then computed an embedding of H* into G, with a relatively small number of fake edges. In
our last step, we compute a large subset S of vertices of G such that G[S] is a v,-expander,
using Algorithm EXTRACTEXPANDER from Lemma [8.2.6) We now proceed to describe each
of the three steps in turn. Throughout the algorithm, we use a parameter z = (ﬁgéz, where
c is a large enough constant, whose value will be set later.

Step 1: Embedding Many Small Expanders. The goal of this step is to construct
a collection H = {H,,..., Hg} of expanders, where for 1 < i < N, V(H;) = V;, and to
compute an low-congestion embedding of all these expanders into G + F', where F' is a small
set of fake edges for G. In other words, if we let H be the graph obtained by taking a
disjoint union of the graphs H;,..., Hy, and the set Z of isolated vertices, then we will
attempt to compute an embedding of H into G. We will either find such an embedding,
that uses relatively few fake edges, or we will return a cut (X,Y) of G with the required
properties. We summarize this step in the following lemma.

Lemma 8.4.5

There is a deterministic algorithm that either computes a cut (X,Y) in G with
X, Y] > Q (‘i’;’;:) and ¥o(X,Y) < 1/100; or it constructs a collection H =

{Hy,...,Hg} of -expanders, where for 1 < i < N, V(H;) = V;, and ¢ = Ye-1/2,
together with a set F' of at most O(zlogn) fake edges, and an embedding P of the
graph H = (|J, H;) U Z into G + F, with congestion O(log” n), such that every vertex
of G is incident to at most O(logn) edges of F'. The running time of the algorithm is
O (N9t - polylogn) + O (N -logn) - T(q — 1).
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Proof. The construction of the graphs Hi,..., Hy, and of their embedding into G is done
gradually, by running N instances of the cut-matching game, in parallel. Initially, for each
1 < i < N, we let the graph H; contain the set V; of vertices and no edges. Throughout
the algorithm, we denote by H = {H, ..., Hyg} the current collection of the expanders we
are constructing. We partition H into two subsets: set H' of active graphs, and set H” of
inactive graphs. Initially, every graph H; is active, so H' = H and H” = (). Throughout the
algorithm, for every inactive graph H;, we will maintain a subset S; C V; of at least N’ /2
vertices, such that graph H;[S;] is a 1,_;-expander. Throughout the algorithm, we also let
H denote the graph obtained by taking the disjoint union of all graphs in ‘H with a set Z of
isolated vertices. We will maintain an embedding P of H into G throughout the algorithm.
We will ensure that, throughout the algorithm, for all 1 < i < N, the maximum vertex
degree in each graph H; is at most ccyg log N

At the beginning of the algorithm, for each 1 < ¢ < N, graph H, contains the set V; of
vertices and no edges, so graph H consists of the set V' of vertices and no edges. The initial
embedding is P = (), and every graph H; is active.

As long as H' # ), we perform iterations, where the jth iteration is executed as follows.
We apply algorithm A(q — 1) to every graph H; € H' separately. Observe that each such
graph contains N’ < Nt vertices, and has maximum vertex degree at most cqcyg log N
For each such graph H;, if the outcome is a subset S; C V; of vertices, such that |S;| > N’ /2
and H;[S;] is a ¢,_;-expander, then we add H; to the set " of inactive graphs, and store the
set S; of vertices with it. Let H C H’ be the collection of all remaining active graphs, so for
each graph H; € #, the algorithm has computed a cut (A4;, B;) with |A;],|B;| > N'/4, and
|Ey, (A, B;)| < N'/100. We assume without loss of generality that |A;] < |B;|. Let (A%, B!)
be any partition of V; with |A}| = |Bi|, such that A; C Al. We treat the partition (A}, B})
as the move of the cut player in the cut-matching game corresponding to the graph H;.

For convenience, we assume w.l.o.g. that H = {Hy,...,Hy}. In order to implement the
response of the matching player, we apply Algorithm ROUTEORCUT from Corollary to
graph G, the sets

Al By, ... Ay, By, of vertices, and parameters ¢ = 1/100 and z (recall that we have de-
fined z = % for some large enough constant ¢). We now consider two cases. If Algorithm
ROUTEORCUT from Corollary returns a cut (X,Y) of G with |X]|,|Y| > z/2 and
Ue(X,Y) < 9, then we say that the current iteration terminates with a cut. In this case,
we terminate the algorithm, and return (X,Y’) as its outcome; it is immediate to verify
that this cut has the required properties. In the second case, we obtain a partial routing
(M = U?Zl M!,P') of the sets A", B, ..., A, B, of vertices, where |M’| > kN'/2 — z (re-
call that for all 4, |4}| = |V;|/2 = N’/2). The congestion of the embbedding is at most
O(A%log® n/v?) = O(log*n). We then say that the current iteration has terminated with a
routing.

Consider now some index 1 < i < k, and let A7 C A. and B! C B! be the subsets
of vertices that do not participate in the matching M. Let M/ be an arbitrary perfect
matching between the vertices of A and the vertices of B/, and let F; be a set of fake edges
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F, = {(u,v) | (u,v) € M!"}. For every pair (u,v) € M/, we embed the pair (u,v) into the
corresponding fake edge (u,v) € F;. Let M/ = MU M. We add the edges of M to graph
H;.

Denote M7 = Ule Mg, and let FV = Ule F; be the resulting set of fake edges; recall
that |F7| < z. Let P7 be the embedding of all edges in M7 that is obtained from the partial
routing P’, by adding the embeddings of all fake edges to it. Observe that we have now
obtained an embedding P? of all edges of M’ into G + F7, with congestion O(log*n). We
add the paths of P7 to the embedding P of the current graph H, and continue to the next
iteration.

Our algorithm can therefore be viewed as running N parallel copies of the cut-matching
game. From Theorem , the number of iterations is bounded by coue log N’, and so for
every graph H;, its maximum vertex degree is always bounded by ccyc log N’. The algorithm
terminates once all graphs H; become inactive. Recall that for each such graph H;, we are
given a subset S; of its vertices, such that |S;| > |Vi|/2, and H;[S;] is a 1,—1-expander. We
perform one last iteration, whose goal is to turn each graph H; into an expander, by adding a
new set of edges to it, while simultaneously embedding these edges into the graph G together
with a small number of fake edges, or find a cut (X,Y) as required. Let r — 1 denote the
index of the last iteration before every graph H; becomes inactive.

Last Iteration. For each 1 < i < N, we let B; = S; and A; =V, \ S;, so that |A4;| <
| B;| holds. We apply Algorithm ROUTEORCUT from Corollary to graph G, the sets
Ay, By, ..., Ay, By of vertices, and parameters ¢» = 1/100 and z. The remainder of the
iteration is executed exactly as before. If Algorithm ROUTEORCUT from Corollary
returns a cut (X,Y) of G with |X|,|Y| > 2/2 and Vs(X,Y) < 4, then we terminate the
algorithm and return this cut. Otherwise, we obtain a partial routing (M’ = U~ , M/, P’)
of the sets Ay, By, ..., Ag, By of vertices, where |M’| > S°N |A;| — z, whose congestion is
at most O(log" n) as before.

Consider some index 1 < i < N, and let A, C A; and B! C B; be the subsets of vertices
that do not participate in the matching M. Let M/ be an arbitrary matching, in which every
vertex of A} is matched to some vertex of Bj, and let F; be a set of fake edges corresponding
to this matching M/, defined as before. For every pair e = (u,v) € M/, we embed the fake
edge e into the path P(e) = (e). Let M] = M]U M. We add the edges of M to graph H;.

Denote M" = Ufil M, and let F" = Ufil F;; be the resulting set of fake edges; as before
|F"| < z. Let P" be the embedding of all edges in M" that is obtained from the partial
routing P’, by adding the embeddings of all fake edges to it. As before, we have obtained an
embedding P” of all edges of M" into G + F", with congestion O(log* n). We add the paths
of P" to the embedding P of the current graph H. It is not hard to see that every graph
H; € H is now a 1,1 /2-expander.

Recall that the congestion incurred by each path set P7 of edges is O(log* n), and, since
the number of iterations is O(logn), the embedding P causes congestion O(log”n). The
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total number of fake edges in F' = U§:1 F7is O(zlogn). Since each set FV of fake edges is
a matching, every vertex of G is incident to O(logn) fake edges.

We now analyze the running time of the algorithm. As observed before, the algorithm
has O(logn) iterations. In every iteration, we apply algorithm A(¢ — 1) to N = O(N)
graphs. Additionally, we use Algorithm ROUTEORCUT from Corollary whose running

time is O(klE(G)|/@/)3 + kn/y?) = O(kn), where k is the number of vertex subsets. Since
k < |H|= N < N, this running time is bounded by O(Nn) = O(N®*"). Therefore, the total
running time of the algorithm is O (N9™') + O (Nlogn) - T(q — 1).

]

Step 2: Embedding One Large Expander. We use Lemma[8.2.1]to construct, in time
O(N), a¢*-expander H' = H on N vertices, with ¢/* = U(H') = Q(1), such that maximum
vertex degree in H' is at most 9. For convenience, we denote V(H') = {vy,...,v5}. The
main part of this step is summarized in the following lemma.

Lemma 8.4.6

There is a deterministic algorithm, that either computes a cut (X,Y) in G with
| X, Y] > Q (M) and Yo (X,Y) < 1/100; or it computes a collection F’ of at

log®n
most 17z fake edges in G, and, for every edge e = (v;,v;) € E(H') a set P(e) of N’
paths in G + F’, such that every path in P(e) connects a vertex of V; to a vertex of
V;, and the endpoints of the paths in P(e) are disjoint. Moreover, every vertex of G
is incident to at most 17 fake edges in F’, and every edge of G U F’ participates in at
most O(log® n) paths in Ucep(mr P(€). The running time of the algorithm is O (NeH).

\. J

Proof. Using a standard greedy algorithm, and the fact that the maximum vertex degree in
H'’ is at most 9, we can partition the set E(H') of edges into 17 matchings, My, ..., M.
We then perform up to 17 iterations; in each iteration ¢, we will either compute a small set
F' of fake edges for G, and the sets P(e) of paths for all edges e € M;, in graph G + F*, or
we will compute the cut (X,Y) in G with the required properties.

In order to execute the ith iteration, we consider the set M; of edges of H’', and denote,
for convenience, M; = {ey,..., ey }. Foreach 1 < j < k;, if e; = (v,,v,), then we define
A; = V., and B; = V... Observe that |4;] = |B;| = N, and the resulting vertex sets
Ay, By, ..., Ay, By, are all disjoint.

We apply Algorithm ROUTEORCUT from Corollary [8.3.7] to graph G, the sets
Ay, By, ..., Ay, By, of vertices, and parameters ¢» = 1/100 and z (as defined before, z =
5)12}7;) If Algorithm ROUTEORCUT returns a cut (X,Y) of G with |X|,|Y] > z/2 and
Ue(X,Y) < 1), then we terminate the algorithm and return this cut; it is easy to verify
that cut (X,Y’) has all required properties. In this case we say that the iteration termi-
nates with a cut. Otherwise, we obtain a partial routing (M; = |J,. M, M(e), P;) of the sets

. ~ k; . .
A1, By, ..., Ay, By, of vertices, where [M;[ > > 71, |A;| — 2, whose congestion is at most
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O(A%log® n/v?) = O(log* n). In this case we say that the iteration terminates with a rout-
ing. Consider now some edge e; € M;. Let Ag. C A, B; C Bj; be the subsets of vertices
that do not participate in the matching M(e;). Let M’(e;) be an arbitrary perfect match-
ing between A’ and B}, and let F]'L be the corresponding set of fake edges for graph G (so
for every edge e € M / (ej), we add an edge with the same endpoints to F;) Finally, set
M"(e;) = M; U ]V[J’ Let F' = U;“:l F}; recall that |[F'| < z. Let P} be the set of paths
routing the edges of F*, where for each edge ¢ € F*, the corresponding path P(e) € P!
consists of the edge e. Lastly, let P/ = P; UP!. Note that P/ is the routing of all edges in
Mi” U F% in graph G + F*, that causes edge-congestion at most O(log4 n).

If any iteration of the algorithm terminated with a cut, then we terminate the algorithm
and return the corresponding cut. We assume from now on that every iteration of the
algorithm terminated with a routing. Setting F’ = Uzl; F', we obtain the desired routing
of the edges of H' in graph G + F’, with congestion O(log® n). Since, for every 1 <i < 17,
the edges of F' form a matching, every vertex of G is incident to at most 17 such edges.

Recall that the running time of Algorithm ROUTEORCUT from Corollary is
O(K|E(G)|/4? + kn/¢?) = O(kn) = O(kN1), where k is the number of pairs of sets that we
need to route. Since k < |V(H')| < N < O(N), and the number of iterations is at most 17,
we get that the running time of the algorithm is O (N¢1). O

Finally, we need the following claim, in order to connect the set Z of extra vertices to
the remaining vertices of G.

Claim 8.4.7

There is a deterministic algorithm, that either computes a cut (X,Y) in G with
| X[, |Y] > Q <¢q 1") and Ve (X,Y) < 1/100; or it computes a matching M con-
necting every vertex of Z to a distinct vertex of V(G) \ Z, a collection F” of at most
z fake edges in G, and a set P” = {P(e) | e € M} of paths in G + F”, such that, for
each edge e = (u,v) € M, path P(e) connects u to v. Moreover, every vertex of G is
incident to at most one fake edge in F”, and every edge of G U I participates in at
most O(log*n) paths in P”. The running time of the algorithm is O (N).

Proof. We apply Algorithm ROUTEORCUT from Corollary[8.3.7to graph G, the sets A; = Z
By = V(G) \ Z of vertices, parameter ¥ = 1/100, and parameter z. If the outcome of
Algorithm ROUTEORCUT is a cut (X,Y) of G with | X]|, |Y| > 2/2 and V(X,Y) < 1), then
we return this cut; it is immediate to verify that cut (X,Y’) has the required properties.
Otherwise, we obtain a routing (M’,P’) of the sets Aj, By, with |M'| > |Z| — z. The
congestion of the routing is at most O(A?log®n/¢?) = O(log*n). We let Z' C Z be the
set of all vertices of Z that do not participate in the matching M’, and we let M” be an
arbitrary matching that matches every vertex of Z’ to a distinct vertex of V(G) \ Z, such
that M = M’ U M" is a matching; such a set M" exists since Z contains at most half the
vertices of G. We let F” be a set of fake edges for G corresponding to the edges of M", so
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every edge e = (u,v) € M" is also added to F". We let P(e) be the path that only consists
of the edge e, and we treat P(e) as the embedding of e.

We now obtained a set F” of at most z fake edges, and every vertex of G is incident
to at most one such fake edge. We also obtained an embedding P” = P' U {P(e) | e € F"}
of M into G with congestion O(log*n). The running time of Algorithm ROUTEORCUT is
O(A%|B(G)|/4° +n/y?) = O(n) = O(NY). O

If the algorithm from Lemma or the algorithm from Claim[8.4.7produce a cut (X, Y)
in G with | X|[,|Y] > Q <M> and Ve (X,Y) < 1/100, then we terminate the algorithm

log® n
and return this cut. Othergwise, consider the following graph H*: we start by letting H*
be a disjoint union of the graphs Hy, ..., Hy constructed in the first step. Additionally, for
every edge e = (v;,v;) € E(H'), for every path P € P(e), whose endpoints are z € V;,
y € V;, we add the edge (z,y) to E(H*). It is immediate to verify that graph H* is an
N’-composition of Hy,...,Hg, and graph H'. Recall that for all 1 < ¢ < N, graph H, is
a 1,_1/2-expander, while graph H’ is a ¢*-expander, for some ¢* = €}(1). The maximum
vertex degree in H' is bounded by 9. Therefore, from Theorem [8.4.4] graph H* is a '
expander, for ¢ = 1¢,_19*/O(logn) = Q(1,—1/logn). Note that the maximum vertex
degree in H* is O(logn). Lastly, we add to graph H* the set Z of extra vertices as isolated
vertices, and the matching M that was computed in Claim [.4.7 Observe that graph H*
is a ¢ /2-expander, where ¢ = Q(,_1/logn); to simplify the notation, we say that H* is
a 1)'-expander, adjusting the value of 9" accordingly. Let F* = F'U F’' U F” be the union
of the sets of fake edges computed by the algorithms from Lemma [8.4.5 Lemma and

Claim [8.4.7| Recall that |F*| = O(zlogn), where z = ;%;% for some large enough constant
c.

We denote by Ag the maximum vertex degree of G + F*. Since the set F™* of fake edges
consists of O(logn) matchings, Ag = O(logn).

By combining the outcomes of the algorithms from Lemma [8.4.5] Lemma [8.4.6] and
Claim , we obtain an embedding of H* into G + F* with congestion at most O(log” n).
The maximum vertex degree in H*, that we denote by Ay, is O(logn). The maximum
vertex degree in G + F*, that we denote by Ag, is O(logn). Note that the running time of
the algorithm so far is O (N7™! - polylogn) + O (N -logn) - T(q — 1).

Step 3: Obtaining the Final Expander. In this step, we apply Algorithm EXTRACT-
EXPANDER from Lemma to graphs G and H*, the set F* of fake edges, and the
embedding of H* into G + F* with congestion at most = O(log® n). We need first to verify
that |F*| < 4" Recall that ¢/ = Q(¢,_1/logn), Ag = O(logn), and n = O(log® n).

32Aagn

Therefore, 32%/’;” > Q (g—%), while |F*| < O(logn) - cwl((l);érrll Setting the constant ¢ to be

large enough, we can ensure that the inequality indeed holds.
Recall that Algorithm EXTRACTEXPANDER from Lemma [8.2.6] computes a subgraph

G' C G, that is a ¢)"-expander, for ¢" > & =0 (wq’l >, as 1" = Q(1,—1/logn). Recall

log” n
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also that 1, 1 = 1/((¢ — 1) log N)8¢=Y and n < N9. Therefore:

1 1 1 -
ce (((q — log N0 - (glog N)?) = (qlog Nt 17

Note that the number of vertices in G’ is at least: n — ‘”Z—t‘". Since %—T'" <0 (M)

"/}qfl
and z = zﬁgéz, letting ¢ be a large enough constant, we can ensure that |V (G')| > 2n/3, as

required.

The running time of Algorithm EXTRACTEXPANDER is O(|E(G)|Ag-n/v") = O(n/1y-1)
= O(N? - (qlog N)™).

By combining all three steps together, we obtain total running time
O (N7 (qlog N)8+OM) + O (N -logn) - T(q — 1), as required.

8.5 A Slower Algorithm for BalCutPrune

In this section we prove the following:

Theorem 8.5.1

There is a universal constant ¢, and a deterministic algorithm, that, given an n-vertex
m-edge graph G = (V, E'), a parameter 0 < ¢ < 1, and another parameter r < clogm,
returns a cut (A4, B) in G with |Eg(A, B)| < ¢ - vol(G), such that:

e cither volg(A), volg(B) > vol(G)/3; or
* volg(A) > & - vol(G), and the graph G[A] has conductance ¢/ > ¢/ 1og®") m.
The running time of the algorithm is O (m1+0(1/r) - (log m)O(TQ)/(bQ).

From the definition of the BalCutPrune problem from Definition [8.1.1] this implies a
slower version of Theorem [8.1.2] when the conductance parameter ¢ is low:

Corollary 8.5.2: Deterministic BalCutPrune algorithm

There is a deterministic algorithm, that, given a graph G with m edges, and parameters
¢ € (0,1],1 <r < O(logm), and o = (logm)°"), computes an a-approximate solution
to instance (G, ¢) of BalCutPrune in time O <m1+0(1/7") - (log m)O(TQ)/qb?).

While the above algorithm can significantly slower than the one from Theorem [8.1.2
when the conductance parameter ¢ is low, many of our applications only need to solve the
BalCutPrune problem for relatively high values of ¢, and so the algorithm from Theorem8.5.1
is sufficient for them. In particular, we will use this algorithm in order to obtain fast
deterministic approximation algorithms for max s-t flow, which will then in turn be used in
order to obtain the full proof of Theorem The remainder of this section is dedicated
to the proof of Theorem [8.5.1]
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Two key ingredients in the proof are an extension of Theorem to smaller sparsity
regime, and a degree reduction procedure, that are discussed in the next two subsections,
respectively.

8.5.1 Extension of Theorem [8.1.3| to Smaller Sparsity

The algorithm from Theorem only guarantees to find a cut with sparsity at most 1/25.
In this subsection, we show an extension of Theorem that is given a target sparsity
parameter ¢ (which can be much smaller than 1/25), and if the algorithm returns a cut,
then that cut has sparsity at most :

Lemma 8.5.3

There is a deterministic algorithm, that, given an n-vertex graph G = (V, F), with
maximum vertex degree A, parameters 0 < ¢ < 1, z > 0 and r > 1, such that
n'/" > Ny (where Ny is the constant from Theorem [8.1.3)), returns one of the following:
e cither a cut (X,Y) in G with | X|[,|Y]| > z/A and ¥4(X,Y) < 9; or
e a graph H with V(H) = V(G), that is a v,(n)-expander (for i.(n) =
1/(logn)°™), together with a set F' of at most O(zlogn) fake edges for G,
and an embedding of H into G + F with congestion at most O(Alogn/v), such
that every vertex of G is incident to at most O(logn) edges of F'.

The running time of the algorithm is O <n1+0(1/r) - (log )0 + HAQ/’Q/J).

Proof. 1f the number of vertices in graph G is odd, then we add an additional new vertex
vp, and we connect it to an arbitrary vertex of G with a fake edge. For simplicity, the new
number of vertices is still denoted by n.

Our algorithm runs the cut-matching game, as follows. We start with a graph H, whose
vertex set is V', and whose edge set is empty, and then perform iterations. Throughout the
algorithm, we will ensure that the maximum vertex degree in H is O(logn).

Iteration i is executed as follows. We apply Algorithm CUTORCERTIFY from Theo-
rem [8.1.3] to graph H. We now consider two cases. In the first case, the outcome is a cut
(A;, B;) in H, with |4,|,|B;| > n/4 and |Ey(A;, B;)| < n/100. Let (A%, B!) be any partition
of V-with A; C A, B; C Bi, and |A}| = |B}|. We apply Algorithm ROUTEORCUT-1PAIR
from Theorem to graph G, with the vertex sets A}, B/, and parameters z and . If the
outcome is a cut (X,Y) in G with | X|, |Y| > z/A and V4(X,Y) < ¢, then we terminate
the algorithm and return this cut as its outcome. Otherwise, we obtain a partial routing
(M;,P;) of the sets A}, Bl, of value at least |A}| — z, that causes congestion at most 4A /.
Let AY C Al B! C B! be subsets of vertices that do not participate in the matching M;. Let
M! be an arbitrary perfect matching between A7 and B/, and let F; be a set of fake edges
corresponding to the matching M/ (so every edge in the matching becomes a fake edge).
For every edge e € F;, we also let P(e) be a path consisting of only the fake edge e. Let
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M!" = M; UM/, and let P, = P, U{P(e) | e € F;}. Then M/ is a perfect matching between
Al and B, and P/ is a routing of this matching in G U F;, with congestion at most 4A /.
We add the edges of M/ to H, and continue to the next iteration.

Consider now the second case, where the outcome of Algorithm CUTORCERTIFY from
Theorem is a subset S C V of at least n/2 vertices, such that W(G[S]) > ¥.(n).
Let i* be the index of the current iteration. We then let B;x = S and A;x = V' '\ S; note
that |Ax| < |Bj<| must hold. We again employ Algorithm ROUTEORCUT-1PAIR from
Theorem with the vertex sets A;-, B;«, and parameters z and 1. If the outcome is a
cut (X,Y) in G with | X|,|Y| > z/A and ¥4(X,Y) < 9, then we terminate the algorithm
and return this cut as its outcome. Otherwise, we obtain a partial routing (M;, P;+) of the
sets A, B+, of value at least |A;«| — z, that causes congestion at most 4A /i). As before, we
let Al. C A+, Bl. C B« be subsets of vertices that do not participate in the matching M;-.
Let M]/. be an arbitrary matching, that matches every vertex of Al. to some vertex of Bl.,
and let Fj« be a set of fake edges corresponding to the matching M/.. As before, for every
edge e € I+, we let P(e) be a path consisting of only the fake edge e. Let M/l = M U M.,
and let P.. = Pi» U{P(e) | e € F}. Then M/, matches every vertex of A;» to a distinct
vertex of B;«, and P is a routing of this matching in GU F}«, with congestion at most 4A /).
We add the edges of M. to H, and terminate the algorithm.

Observe that, if the algorithm never terminates with a cut (X,Y) with | X|,|Y] > z/A
and Ve (X,Y) < 9, then the final graph H is a 1,.(n)/2-expander. Moreover, if we let
F = UZ:1 F;, together with an additional fake edge incident to vg if the initial number of
vertices in G was odd, and P = UZ:1 P/, then P is an embedding of H into G + F. From
Theorem , the number of iterations in the algorithm is bounded by O(logn). Since, for
all i, edge set F; is a matching, every vertex of G is incident to O(logn) edges of F. Since
every set F; contains at most z edges, |F| = O(zlogn). Lastly, since every set P; of paths
causes congestion O(A/v), the paths in P cause congestion O(Alogn/¢). It now remains
to bound the running time of the algorithm.

The algorithm performs O(logn) iterations. Each iteration requires running the Algo-

rithm CUTORCERTIFY from Theorem [8.1.3) which takes time O (n”o(l/”) - (log n)o(r2)>,

and Algorithm ROUTEORCUT-1PAIR from Theorem [8.3.8 that takes time O (nA2/v).
Therefore, the total running time of the algorithm is: O (n!'+001/7) . (logn)°*) + nAQ/w).
L]

8.5.2 Degree Reduction

Assume that we are given a graph G = (V| E) with |V| = n and |E| = m, that we view as an
input to the BalCutPrune problem. In this subsection we show a deterministic algorithm, that
we call REDUCEDEGREE, that has running time O(m), and transforms G into a bounded-
degree graph G. We also provide an algorithm that transforms any sparse balanced cut in a
subgraph of G into a “nice” cut, that corresponds to a sparse balanced cut in a subgraph of
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G.

We first describe Algorithm REDUCEDEGREE for constructing the graph G. For conve-
nience, we denote V' = {vy,...,v,}. For every vertex v; € V', we let deg(v;) denote the degree
of v; in G, and we let {61(01-), . ,edeg(vi)(vi)} be the set of edges incident to v, indexed in
an arbitrary order. For every vertex v; € V, we use Lemma to construct a graph H;
on a set V; of deg(v;) vertices, that is an ap-expander, for some constant o, such that the
maximum vertex degree in H; is at most 9. Recall that the running time of the algorithm for
constructing H; is O(deg(v;)). We denote the vertices of H; by Vi = {u1(v;), .. ., Udeg(w;)(v3) }-

In order to obtain the final graph G, we start with a disjoint union of all graphs in
{H; | v; € V}. All edges lying in such graphs H; are called type-1 edges. Additionally, we
add to G a collection of type-2 edges, defined as follows. Consider any edge e = (v,v') € E,
and assume that e = e;(v) = e;(v') (that is, e is the jth edge incident to v and it is the j'th
edge incident to v'). We then let é be the edge (u;(v),uj(v)). For every edge e € E, we add
the correspondmg new edge é to graph G as a type-2 edge. This concludes the construction
of the graph G, that we denote by G = (V, E). Note that the maximum vertex degree in G
is at most 10, and |V| = 2m. Moreover, the running time of the algorithm for constructing
the graph G is O(m).

We say that a subset S C V of vertices is canonical iff for every vertex v; € V, either
V; €S, or V;NS = . Similarly, we say that a cut (X,Y") in a subgraph of G is canonical
iff each of X,Y is a canonical subset of V. The following lemma allows us to convert an
arbitrary sparse balanced cuts in a subgraph of G into a canonical one.

Lemma 8.5.4

Let ap > 0 be the constant from Lemma [8.2.1] There is a deterministic algorithm,
that we call MAKECANONICAL, that, given a subgraph G/ C G, where V(G’) is a
canonical vertex set, and a cut (A4, B) in G’, computes, in time O(m), a canonical cut
(A, B") in (', such that |A’| > |A|/2, |B'| > |B|/2, and moreover, if |E4(A, B)| <
Ymin {|A], |B|}, for ¥ < ap/2, then |E4(A’, B")| < O(|E4(A, B))).

. J

Proof. We start with the cut (A, B) = (4, B) in graph G’ and then gradually modify it, by
processing the vertices of V(G) one-by-one. When a vertex v; is processed, if V; NV (G') # 0,
we move all vertices of V; to either A or B. Once every vertex of V(G) is processed, we
obtain the final cut (A’, B’), that will serve as the output of the algorithm.

Consider an iteration when some vertex v; € V(@) is processed, and assume that V; C
V(G'). Denote A, = ANV; and B; = BNV, If |A] > |B;|, then we move all vertices
of B; to A, and otherwise we move all vertices of 4; to B. Assume w.lo.g. that the
latter happened (the other case is symmetric). Note that the only new edges that are
added to the cut Eg(A, B) are type-2 edges that are incident to the vertices of A;. The
number of such edges is bounded by |A;|. The edges of Ey,(A;, B;) belonged to the cut
EG(A, B) before the current iteration, but they do not belong to the cut at the end of
the iteration. Since H; is an ag-expander, we get that |A;| < |Eg,(A;, B;)|/ap. Therefore,
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the increase in |Eg(A, B)|, due to the current iteration is bounded by |Eg,(A;, Bi)|/ao.
We charge the edges of Ey,(A;, B;) for this increase; note that these edges will never be
charged again. The algorithm terminates once all vertices of V(G) are processed. Let
(A, B') denote the final cut (A, B). From the above discussion, we are guaranteed that
Ea(A, B < |E(A B)|+ X, v ey | B (Ai, Bl /oo < O(Eg(A, B))).

Next, we claim that |A’| > |A|/2 and that |B’| > |B|/2. We prove this for |A’|; the proof
for |B'| is symmetric. Indeed, assume otherwise. Let V' C V' be the set of all vertices Vs,
such that, when the algorithm processed v;, the vertices of A; were moved from A to B,
and let n; = [A;]. Then »_ i/ n; > [A]/2 must hold. Notice however that for a vertex

e V' |Eg, (A, Bi)| > ao|A;| = agn; must hold. Therefore, graph H; contributed at least
aon; edges to the original cut E4(A, B). Since we are guaranteed that |Exs(A, B)| < - |A],
we get that > . aon; < ¢ - |A], and so Y, ovni < Y- [Al/ag < |A]/2, since we have
assumed that ¢ < ag/2. But this contradicts the fact that we established before, that
Y ovevr i > |A]/2. O

8.5.3 Completing the Proof of Theorem [8.5.1

We prove the following theorem, from which Theorem [8.5.1 immediately follows.

Theorem &.5.5

There is a universal constant N, and a deterministic algorithm, that, given an n-vertex
m-edge graph G = (V, E), a parameter 0 < ¢ < 1, and another parameter r > 1, such
that m!/" > N, returns a cut (A, B) in G with |Eg(A, B)| < ¢ - vol(G), such that:

e cither volg(A), volg(B) > vol(G)/3; or

e volg(A) > L - vol(G), and the graph G[A] has conductance ¢/ > ¢/log®" m

The running time of the algorithm is O ( 1+O(1/7) . (log m)°C* /¢2>

In order to complete the proof of Theorem [8.5.1 we let ¢ be a large enough constant, so
that m!/(cloem) > N{ holds. We then apply the algorithm from Theorem m to the input
graph G and the parameter r. In the remainder of this section we focus on the proof of
Theorem R.5.5
Proof of Theorem “ We denote by v, (n) = 1/10g?" n the parameter from Theo-
rem (that is, when Algorithm CUTORCERTIFY from Theorem returns a set S
of at least n/2 vertices, then W(G|[S]) > ¢,(n) holds). Throughout the proof, we use two
parameters: ) = ¢/¢, and z = é(loz—:rvlz)é’“’ where ¢ is a large constant to be set later. We also
set V) = 4Ny, where Ny is the universal constants from Theorem m

We start by using Algorithm REDUCEDEGREE described in Section [8.5.2] in order to
construct, in time O(m), a graph G whose maximum vertex degree is bounded by 10, and
[V(G)| = 2m. Denote V(G) = {vy,...,v,}. Recall that graph G is constructed from graph
G by replacing each vertex v; with an ag-expander H; on deg(v;) vertices, where ag = O(1).
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For convenience, we denote the set of vertices of H; by V;. Therefore, V(G) is a union of the
sets Vi,...,V, of vertices. Consider now some subset S of vertices of G. Recall that we say
that S is a canonical vertex set iff for every 1 < i < n, either V; C S or V; NS = () holds.

The algorithm performs a number of iterations. We maintain a subgraph G CG ; at the
beginning of the algorithm, &' = G. In the ith iteration, we compute a canonical subset
S; C V(G’ ) of vertices, and then update the graph G’, by deleting the vertices of S; from it.
The iterations are performed as long as ||, Si| < [V(G)]/3.

In order to execute the ith iteration, we consider the current graph G’, denoting |V(é’ )| =
n'. Note that, since we assume that |{J,_, S¢| < IV(G)|/3, we get that n' > 2|V(G)|/3.
From our choice of parameter N, we are guaranteed that (n/)}/" > N;. We can now apply
Lemma m to graph G , with the parameters r, ¢ and z. Recall that the maximum
vertex degree in G is A < 10. Assume first that the outcome is a cut (X,Y) in G’ with
| X[, Y] > 2/A > 2/10 and V4, (X,Y) < ¢. We say that the iteration terminates with a
cut in this case. By setting ¢ to be a large enough constant, we can ensure that ¢ < /2
where o is the constant from Lemma [8.2.1f We use the algorithm MAKECANONICAL from
Lemma m to compute, in time O(m), a canonical partition (X’,Y”) of V(G’), such that
| X', |Y'] > Q(2), and |Eg (X', Y")| < O(|Ez/(X,Y)]). Assume w.lo.g. that | X’| <|Y'|. We
are then guaranteed that | X'| > €2(2), and that for some constant p, |Ez (X', Y")| < pp| X',
or equivalently, W, (X', Y') < up. We set S; = X', delete the vertices of S; from G,
and continue to the next iteration. Observe that set V(G’ ) of vertices remains canonical.
Otherwise, the outcome of Lemma is a graph H with V(H) = V(&), that is a ¢, (n)-
expander, together with a set F' of at most O(zlogn) fake edges for (', and an embedding of
H into & + F with congestion at most O(logm /1), such that every vertex of G/ is incident
to at most O(logm) edges of F. In this case we say that the iteration terminates with an
expander. If an iteration terminates with an expander, then the whole algorithm terminates.

Let 7 denote the index of the last iteration of the algorithm that terminated with a cut.
Recall that one of the following two cases must hold:

e (Case 1): the algorithAm had exactly ¢ iterations, every iteration terminated with a cut,
and ’Ui’gi Syl = [V(G)|/3;

e (Case 2): the algorithm had (i + 1) iterations, the first ith iterations terminated with
cuts, and the last iteration terminated with an expander.

In either case, let S = Uz:,:l Sy. Then S is a canonical vertex set for G , and moreover, it
is easy to verify that:

[B(S,5)| < mplS| < puy|V(G)]. (8.1)

Assume first that Case 1 happened. Consider the partition (A’, B") of V(G), where
A" = S and B = V(G)\ S. Recall that | Uy Sir| < IV(G)|/3 held (or we would not
have executed the ith iteration). Let G denote the graph G’ that served as input to the
ith iteration, and let n; = |V(G;)|. Then n; > 2|V(G)|/3. Let (X;,Y;) be the cut that was
returned by Lemma , and let (X!, Y7) be the canonical cut that we obtained in G/, so that

(]
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S; = X!. Recall that | X!| < |Y/]. Tt follows that |Y/| > [V(G)|/3, and | U, -, S¥| > [V(G)]/3.
Since A’ = J, -, Sy and B’ =Y/, we get that |A'|,|B'| > [V(G)|/3. From Equation ,
Eg(A, B)| < oplV(C)].

Lastly, we obtain a cut (A, B) of V(G) as follows. For every vertex v; € V(G), if V; C A’,
then we add v; to A, and otherwise we add it to B. Since, for every 1 <i < n, |V;| = degq(v;),
it is easy to verify that vol(A) = |A’| > |V(G)|/3 = vol(G)/3, and similarly vol(B) >
vol(G)/3. It is also immediate to verify that |Eq(A, B)| = |Es(A", B')| < )| V(GQ)| =
- vol(G). Since 1p = ¢/¢, by letting ¢ be a large enough constant, we can ensure that
|Eq(A, B)| < ¢-vol(G). We return the cut (A, B) as the outcome of the algorithm.

Assume now that Case 2 happened. Let CA?Z»H denote the graph G’ that served as input
to the last iteration. Recall that in this last iteration, the algorithm from Lemma [8.5.3
returned a graph H with V/(H) = V(Gi41), that is a ¢, (n')-expander, where n’ = [V (G4q| >
2|V (G)|/3, together with a set F of at most O(zlog n) fake edges for G4, and an embedding
of H into Gi11 + F with congestion at most O(logm/1), such that every vertex of Gist is
incident to at most O(logm) edges of F. Let G” be the graph obtained from G, by adding
the edges of F' to it. Then graph H embeds into G” with congestion at most O(logm /1),
and so, from Lemma , graph G is a ¢/-expander, for ¢/ = Q.(n') - ¥/logm) =
Q (¢/(logm)O0).

Recall that all vertex sets St,...,.S; are canonical; therefore, the set V(G” ) of vertices is
also canonical. Let G” be the graph obtained from G” as follows. For every vertex v; € V(G),
if V; C V(@” ), then we contract the vertices of V; into a single vertex v;, and remove all self
loops. Let A" = V(G"). It is easy to verify that G” can be obtained from G[A’], by adding
at most O(zlogm) edges to it — the edges corresponding to the fake edges in F'. Moreover,
vol(A) = [V(G)| —|S] > 2|V(G)|/3 > 2vol(G)/3. Tt is also easy to verify that G” has
conductance at least ¢’. Indeed, consider any cut (X,Y") in G”. This cut naturally defines
acut (X',Y') in G": for every vertex v; € A', if v; € X, then we add all vertices of V; to X’,
and otherwise we add them to Y’. Then |X'| = volg(X) > volg(X), [Y'| = volg(Y) >
volg(Y), and |Ex, (X', Y")| = |Egr(X,Y)|. Since graph G” is a ¢/-expander, we get that
’EGn(X, Y)‘ 2 ’EA,,(X,, Y’)| Z w, min {‘X", ’Y"} 2 w/ min {VOlGn(X), VOIGu(Y)}.

In our last step, we get rid of the fake edges in G” by applying Theorem to it, with
conductance parameter ¢, and the set F' of fake edges; (recall that |F| = O(zlogn), and
z = é(lo‘g—’fn)ér for some large enough constant ¢). In order to be able to use the theorem, we
need to verify that |[F| < '-|E(G")|/10. Since ¢ = Q (¢/(logm)°™)), and |E(G")| > Q(m),
by letting ¢ be a large enough constant, we can ensure that this condition holds. Applying
Theorem to graph G”, with conductance parameter ¢', and the set F' of fake edges, we
obtain a subgraph G’ C G”\ F, of conductance at least ¢ /6 = Q (¢/(logm)°"). Moreover,
if we denote by A = V(G’) and B = V(G")\ V(G"), then |Eg/(A, B)| < 4k and:

volgn(B) < 8k/¢' < O (k- (logm)°" /¢) , (82)

where k = |F| = O(zlogn) is the number of the fake edges. The running time of the
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algorithm from Theorem is O (m/y') = O (m(logm)®"/¢). Let B=V(G)\ A. The
algorithm then returns the cut (A, B). We now verify that the cut has all required properties.
We have already established that G[A] has conductance at least ¢/(logm)°).

Let S = B\ B. Then equivalently, we can obtain the set S C V(G) of vertices from the
set S C V(G) of vertices (recall that S = UL_, Si) by adding to S every vertex v; € V(G)
with V; C S. Since, from Equation (8 ., |Ea(S, S)\ < |V (G)| for some constant s, it is
easy to verify that:

|Ea(S,V(G)\ 8)| < pp - vol(G) = g - vol(G) /. (8.3)

From the above discussion, we are also guaranteed that |Egn (A, B)| < 4|F| < O(z logn).
Since z = (l(g—m)c” by letting ¢ be a large enough constant, we can ensure that | Egr (A, B)| <
¢m /100 < ¢vol(G)/100. Therefore, altogether, we get that:

|E(A, B)| < |Eq(A, B)|+|Ea(S,V(G)\ 8)| < ¢ vol(G)/100+ ¢ vol(G) /& < ¢ - vol(G),

if ¢ is chosen to be a large enough constant.

Lastly, it remains to verify that volg(A) > % - vol(G). Recall that HA/(GZ-H)] >
2/V(G)|/3 > 2vol(G)/3. Therefore, if we denote by U = V(G") = V(G) \ S, then
volg(U) > 2V01( )/3. Recall that A = U \ B, and, from Equation , volgn(B) <
O (k- (logm)® ) O (z - (logm)° ’“)/gb) Moreover, volg(B) < VOlG//(B) + Eq(S,B) <

volG//(B) + Eq(U, S). From Equation (8 , we get that:

volg(B) <O (z - (logm)®"/¢) + O(udvol(G)/¢).

Since z = by letting ¢ be a large enough constant, we can ensure that vols (B

‘JS—C” ) <
¢(logmn)

vol(G)/12. We then get that volg(A) > |V(Gi1)| — volg(B) > 2vol(G)/3 — vol(G) /12 >
7vol(G)/12.

It now remains to analyze the running time of the algorithm. The time required to
construct graph G from graph G is O(m). Recall that, if an iteration terminates with a
cut, then we delete from G/ a set of at least Q(z) vertices. Therefore, the total number of
iterations is bounded by O(|V(G)|/z) = O(m/z) = O ((logm)®™ /). The running time of
each iteration is:

0, <m1+0(1/7") - (logm)°™) + m/w> ( O/ . (logm)O™) + m/¢> :

At the end of each iteration, we employ Lemma to turn the resulting cut into
a canonical one, in time O(m). Therefore, the total running time of the iterations is

o, (m”o(lm - (log m)o(rz)/(b?). Lastly, if Case 2 happens, we employ the algorithm from
Theorem [8.2.3, whose running time, as discussed above, is O (m(logm)°) /). Altogether,
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the running time of the algorithm is O <m1+0(1/” - (log m)()(r2)/¢2>_

8.6 Unweighted Expander Decomposition

Finally, we present the expander decomposition algorithm promised by Theorem m (re-
stated below) using BalCutPrune as a subroutine.

Theorem 8.6.1: Deterministic expander decomposition, unweighted

There is a deterministic algorithm that, given an unweighted graph G = (V| E) and
parameters € € (0,1] and 1 < r < O(logm), computes a (€, ¢)-expander decomposition

of G with ¢ = Q(¢/(logm)®™), in time O (m”OW’”)*O - (log m) (’"2)/62). Setting
r = (logn)'/3, we obtain ¢ = ¢/n°™") and time O(m'+°M) /e?).

Proof. We maintain a collection H of disjoint sub-graphs of G that we call clusters, which
is partitioned into two subsets, set H4 of active clusters, and set H' of inactive clusters.
We ensure that for each inactive cluster H € H!, ®(H) > ¢. We also maintain a set F’
of “deleted” edges, that are not contained in any cluster in H. At the beginning of the
algorithm, we let H = HA = {G}, H! = 0, and E' = (). The algorithm proceeds as long
HA # 0, and consists of iterations. For convenience, we denote o = (logm)” 2, and we set
¢ = ¢/(ca - logm), for some large enough constant ¢, so that ¢ = Q(e/(logm)°) holds.

In every iteration, we apply the algorithm from Corollary - to every graph H € HA,
with the same parameters a, r, and ¢. Consider the cut (A, B) in H that the algorithm
returns, with |Eg (A, B)| < a¢-vol(H) < %;(Z). We add the edges of Ey(A, B) to set E'.
If voly (A), voly(B) > vol(H)/3, then we replace H with H[A] and H[B] in H and in H*.
Otherwise, we are guaranteed that voly(A) > vol(H)/2, and graph H[A] has conductance
at least ¢. Then we remove H from H and H#, add H[A] to H and H!, and add H|[B] to
H and HA.

When the algorithm terminates, H* = ), and so every graph in A has conductance at
least ¢. Notice that in every iteration, the maximum volume of a graph in H“ must decrease
by a constant factor. Therefore, the number of iterations is bounded by O(logm). It is
easy to verify that the number of edges added to set E’ in every iteration is at most w
Therefore, by letting ¢ be a large enough constant, we can ensure that |E'| < evol(G).
The output of the algorithm is the partition P = {V( )| H € H} of V. From the above

discussion, we obtain a valid (e, ¢)-expander decomposition, for ¢ = (e/ (log m)o(’"2)).

It remains to analyze the running time of the algorithm. The running time of a single
iteration is bounded by O <m1+0(1/” - (log m)o(rz)/ﬁ) =0 (mHO(l/T) - (log m)o("Q)/e2).
Since the total number of iterations is bounded by O(logm), we get that the total running
time of the algorithm is O (m”o(lm - (log m)O(TQ)/62>. O
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8.6.1 Spectral Sparsification

Our deterministic algorithm for computing expander decompositions from Theorem [8.6.1
immediately implies a deterministic algorithm for the original application of expander de-
compositions: constructing spectral sparsifiers [L01]. We require this application in the next
section on weighted expander decomposition.

Suppose we are given a undirected weighted n-vertex graph G = (V, E, w) (possibly with
self-loops). The Laplacian Lg of G is a matrix of size n x n whose entries are defined as
follows:

0 u#v, (u,0) ¢ E
Lg(u, U) = —Wqyw u 75 v, (u’ U) cFk
Z(u,u’)GE: Wy u ="n.
uFu/

We say that a graph H is an a-approximate spectral sparsifier for G iff for all x € R",
imTLGa) <az'Lyx <a-z'Lcz holds.

All previous deterministic algorithms for graph sparsification, including those computing
cut sparsifiers, exploit explicit potential function-based approach of Batson, Spielman and
Srivastava [I3]. All previous algorithms that achieve faster running time either perform
random sampling [99], or use random projections, in order to estimate the importances of
edges [5]. We provide the first deterministic, almost-linear-time algorithm for computing
a spectral sparsifier of a weighted graph. We emphasize that although all algorithms from
previous sections are designed for unweighted graphs, the fact that spectral sparsifiers are
“decomposable” allows us to easily reduce the problem on weighted graphs to the one on
unweighted graphs.

Corollary 8.6.2: Deterministic spectral sparsifier

There is a deterministic algorithm, that we call SpectralSparsify that, given
an undirected n-node m-edge graph G = (V, E, w) with integral edge weights w
bounded by U, and a parameter 1 < r < O(logm), computes a (logm)O(TQ)—
approximate spectral sparsifier H for G, with |E(H)| < O (nlognlogU), in time

O (m”o(l/r) - (logm)°™) log U).

Proof. We first assume that G is unweighted. We compute a (1/2, ¢)-expander decomposition
P ={V1,Va,...,Vi.} of G, for ¢ = 1/(log m)O(’"2), using the algorithm from Theorem m
Let F denote the set of all edges e € E(G), whose endpoints lie in different sets in the
partition P. If E # ), then we continue the expander decomposition recursively on G [E]
Notice that the depth of the recursion is bounded by O(log m). When this process terminates,
we obtain a collection {G1,...,G.} of sub-graphs of G, that are disjoint in their edges, such
that (J;_, E(G;) = E(G). Moreover, we are guaranteed that for all 1 < j < z, graph G;
has conductance are at least ¢ = 1/(log m)O(’"2). It is now enough to compute a spectral
sparsifier for each of the resulting graphs G, ..., G, separately.
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We can now assume that we are given a graph G whose conductance is at least ¢ =
1/(log m)O(TQ), and our goal is to construct a spectral sparsifier for GG. In order to do so, we
will first approximate G by a “product demand graph” D, that was defined in [64], and then
use the construction of [64], that can be viewed as a strengthening of Lemma [8.2.1] in order
to sparsify D.

Definition 8.6.3: Product demand graph, Definition G.13 [64]

Given a vector d € (R+)", its corresponding product demand graph H(d), is a com-
plete weighted graph on n vertices with self-loops, where for every pair ¢, j of vertices,
the Welght w;; = dzdj

Given an n-node edge-weighted graph G = (V, E, w), let deg, € Z" be the vector of
weighted degrees of every vertex (that includes self-loops), so for all j € V, the jth entry

of deg is degq(j) = > ;e wiy- Given an input graph G, we construct a product demand

graph D = #(G)H (degy). It is immediate to verify that the weighted degree vectors of D

and G are equal, that is, degp = deg.

Next, we need to extend the notion of conductance to weighted graphs with self loops.
Consider a weighted graph H = (V' E', w’) (that may have self-loops), and let S C V' be a
cut in H. We then let 6 (S) = D (yep. Wi, and we let voly(S) = > oD ey Wi, A

uES,vES
ou(S)
min{volH(S),volH (S)

H is the minimum conductance of any cut in H. We need the following observation:

weighted conductance of the cut S in H is then: , and the conductance of

Observation 8.6.4

The weighted conductance of graph D is at least 1/2.

Proof. Consider any cut S in D. Observe that, from our construction, dp(S) = volg(95) -
volg(S)/vol(G). Tt is also easy to see that voly(S) = volg(S). Assume without loss of

generality that volp(S) < volp(S), so volp(S) > vol(G)/2. Then the conductance of the
cut S is:

5p(S)  vola(S) - vola(3)
volp(S)  vol(G) - volg(S)

1
> —.
-2

In the following lemma, we show that D is a spectral sparsifier for G.

Let D and G be two undirected weighted n-vertex graphs with V(D) = V(G), such
that degp, = deg,. Assume further that ®(D),®(G) > ¢ for some conductance
2

threshold ¢. Then for any real vector & € R™: %wTLGw <z'Lpx < %a)TLGw.
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Proof. The normalized Laplacian Ly of a weighted graph H is defined as ng/ QLHW}}U 2,
where Ly is the Laplacian of H and Wy is a diagonal weighted-degree matrix, where for
every vertex v of H, (Wy),, = degy(v).

Let L p and ZG be normalized Laplacians of D and G, respectively. It is well-known that
eigenvalues of normalized Laplacians are between 0 and 2. Also, observe that, for any graph
H, Ly1 = 0. Therefore, L¢(degy)'/? = Lp(deg)/? = 0. That is, (degy)"/? is in the kernel
of both EG and ZD.

D=

Let A be the second smallest eigenvalue of L. Then for any vector @’ L (degy)?, we
have: \
§£B/TZD$, < )‘HmIHQ < iB/TZGm/,

since the largest eigenvalue of L p is at most 2. This implies that, for every vector & € R",
x' Loz > %:BTLDQZ holds. Indeed, we can write

=7+ c(degG)%

where T L (deg,)? and ¢ is a scalar. This gives:

)

8
—
h
Q
8
I

(E +c (degG)5>T L¢ (E +c (degc)%)

4|
)
Q
8|

>0 >0 > 8
8
=)
S
8

v

T+ c(degg)é)T Lp <E—|— c(degg)%)

S

-z Lpx

where the last equality uses the fact that deg, = degp. By Cheeger’s inequality [6], we have
A > ®(G)?/2 > ¢?/2. Therefore, for any vector ¢ € R™:

2
x Loz > %mTLDa: (8.4)
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We can now conclude that, for any vector € R™:

z' Loz = wTWé/z/L\Gwé/Qw

¢

2
> ZmTWg“LDW};%

2
- %mTWé/QWBI/QLDW,;WWé/Qm

¢* 7
=—x L

1 T DI

where the first inequality follows by applying Equation 1) to vector ' = Wé/ 2:0, and
the last equality follows from the fact that deg, = deg,. The proof that " Lpx > <Z’TQ:ETL HT
is similar.

[]

2
Using Lemma [8.6.5 with ¢ = 1/(logm)°C*) implies that D is a ((logm)O(T2)> =

(log m)o(ﬁ)—approximate spectral sparsifier of H. Finally, a spectral sparsifier for graph
D can be constructed in nearly linear time using the following lemma.

Lemma 8.6.6: Lemma G.15, [64]

There exists a deterministic algorithm that, given any demand vector d € R", com-
putes, in time O(ne™*), a graph K with O(ne™?) edges such that e “K is an e*-
approximate spectral sparsifier of H(d).

By letting € = 2 and d = deg), in Lemma [8.6.6, we obtain an 100-approximate spectral
sparsifier for graph D (by scaling K'), which is in turn a (log m)O(’"2)—approximate spectral
sparsifier for graph G. By combining the spectral sparsifiers that we have computed for all
sub-graphs of the original input graph G, we obtain an (log m)o(”Q)—approximate spectral
sparsifier of the original graph G. The total number of edges in the sparsifier is O(nlogn),
as every level of the recursion contributes O(n) edges.

We now analyze the running time of the algorithm. Since the depth of the recursion is

O(log m), running Theorem 8.6.1|takes O (m”o(l/r) - (log m)O(T2)> time in total. Sparsifying

the resulting expanders takes O(mpolylog(m)) time. Therefore, the overall running time is
bounded by O <m1+0(1/7~) - (log m)O(TZ)).

For the general (weighted) case, it suffices to decompose the graph by the binary repre-
sentations of the edge weights and sum the results up: For every edge e € E(G), let b, be
the binary representation of the weight w,. For all 1 <i < [log(max, w,)], we construct an
unweighted graph G, whose vertex set is V, and edge set contains every edge e € E(G),
such that the ith bit of b, is 1. Since w, < U for every e € F(G), there are at most [log U]
such G™s. By the algorithm for the unweighted case, we compute (log m)O(TQ)-approximate
spectral sparsifiers for each GV, The desired (log m)O(TQ)—approximate spectral sparsifier for
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G is Zlﬂzolg(maxeweﬂ 2iG@. This sparsifier contains 32 »<)l| (G| = O(nlognlogU)

=1
edges. The total running time is O (mHO(l/”) - (logm)°*) log U). O

8.7 Weighted Expander Decomposition with Custom De-
mands

In this section, we present our deterministic algorithm for weighted expander decomposition
with custom demands on the vertices. In this setting, the input graph is weighted and every
vertex v € V(G) has a non-negative demand d(v) that is independent of the edge weights.
We define the demand of a set S C V(G) of vertices is d(S) = ) ¢ d(v). Given a subset
S C V of vertices, we denote by d|s the vector d of demands restricted to the vertices of S.
We start by defining a weighted variant of sparsity and of expander decomposition.

Definition 8.7.1: Weighted sparsity with custom demands

Given a graph G = (V, E) with non-negative weights w(e) > E on its edges e € E,
and non-negative demands d(v) > 0 on its vertices v € V, the d-sparsity of a subset
S C V of vertices with 0 < d(S) < d(V) is:

w(Ee(S,V'\5))

d _
e(S) = min{d(S),d(V \ S)}

The d-sparsity of graph G is V4(G) = mingcy.0<d(s)<d(v) vd(9).

Observe that if w(e) = 1 for all e € E and d(v) = deg(v) for all v € V, then this
definition is exactly the conductance of the graph. Here, we use the term sparsity instead of
conductance because traditionally, sparsity concerns the number of vertices in the denom-
inator of the ratio, while conductance uses volume which is closely related to the number
of edges. However, for lack of an alternative term, we will stick with the term expander to
describe a graph of high weighted sparsity. We now define an expander decomposition for
the weighted sparsity, which generalizes the standard definition for conductance.

Definition 8.7.2: Weighted expander decomposition with custom demands

Given a graph G = (V, E,w,d) with non-negative weights w(e) > 0 on its edges
e € E, and non-negative demands d(v) > 0 on its vertices v € V', a (€,v)-ezpander
decomposition of G is a partition P = {Vq,..., Vi } of the set V of vertices, such that:

1. For all 1 <i <k, the graph G[V;] has d

2. Y5 w(Ea(Ve, V\ Vi) < ed(V).

v;-sparsity at least 1, and

The main result of this section is an algorithm to compute such a decomposition.
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Theorem 8.7.3: Weighted expander decomposition algorithm with custom demands

There is a deterministic algorithm that, given an m-edge graph G = (V, E) with
weights 1 < w(e) < U on its edges e € E, and demands d(v) € {0} U [1,U] for its
vertices v € V that are not all zero, together with a parameter ¢ € (0,1] and r > 1,
computes a (€, ¥)-expander decomposition of G, for ¢ = ¢/ ( logo(’A) mlog U ), in time
m - (mU)°/) log(mU).

8.7.1 Our Techniques

Similarly to the unweighted case, the key subroutine of our expander decomposition algo-
rithm is solving the following WeightedBalCutPrune problem, a generalization of BalCutPrune
(Definition [8.1.1]) that allows both weighted edges and demands on the vertices.

Definition 8.7.4: WeightedBalCutPrune problem

The input to the a-approximate WeightedBalCutPrune problem is a graph G = (V, E)
with non-negative weights w(e) > 0 on edges e € E, a nonzero vector d € R‘z/o of
demands, a sparsity parameter 0 < ¢ < 1, and an approximation factor a. The goal
is to compute a partition (A, B) of V(G) (where possibly B = ), with w(E(A, B)) <
at) - min{d(A),d(B)}, such that one of the following hold: either
1. (Cut) d(A),d(B) >d(V)/3; or
2. (Prune) d(A4) > d(V)/2, and W44 (G[A]) > 1.
We remark that the guarantee w(E(A, B)) < at) - min{d(A),d(B)} is stronger than
what we would obtain if we directly translated BalCutPrune. The latter only requires that
|E(A, B)| < ap - vol(G) in their setting, which would translate to w(E(A, B)) < a1y - d(V)

in our setting.

Theorem 8.7.5: Deterministic WeightedBalCutPrune algorithm

There is a deterministic algorithm that, given an m-edge connected graph G = (V, E)
with edge weights 1 < w(e) < U for all e € E and demands d(v) € {0} U [1, U] for all
v € V that are not all zero, together with parameters 0 < ¢ <1 and r > 1, solves the

(logo(’A) m)-approximate WeightedBalCutPrune problem in time m - (m{)°1/7).

Unlike in the unweighted case, we still do not solve WeightedBalCutPrune directly. Rather,
we reduce it to another problem, the Most-Balanced Cut problem, and then provide a bi-
criteria approximation algorithm for Most-Balanced Cut, this time based on recursively
applying the j-tree framework of Madry [77]. In Section , we then show our algo-
rithm for Weighted Most-Balanced Cut can be used in order to approximately solve the
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WeightedBalCutPrune problem.

Definition 8.7.6: (s, b)-most-balanced -sparse cut

Given a graph G = (V, E) and parameters s,b > 1, a set S C V with d(S) < d(V)/2
is a (s, b)-most-balanced 1-sparse cut if it satisfies:
L w(S,V\S) <v¢-d(9).
2. Define 9* := ¢/s and let S* C V be the set with maximum d(S*) out of all
sets S’ satisfying w(S’, V'\ S") < ¢*-min{d(S"),d(V'\ 5")} and d(S5") < d(V)/2.
Then, d(S) > d(S5*)/b.

Let us first motivate why we consider a completely different recursive framework based
on recursive j-trees [77] instead of the recursive KKOV cut-matching game framework [58]
as used in the unweighted case. This is because KKOV recursion scheme does not generalize
easily to the weighted setting. The main issue that in a weighted graph, the flows constructed
by the matching player cannot be decomposed into a small number of paths; the only bound
we can prove is at most m paths by standard flow decomposition arguments. Hence, the
graphs constructed by the cut player are not any sparser, preventing us from obtaining an
efficient recursive bound. Madry’s j-tree framework, on the other hand, generalizes smoothly
to weighted instances and can even be adapted to solve the sparsest cut problem with general
demands, for which Madry provided efficient randomized algorithms in his original paper [77].

Below, we give a high-level description of Madry’s approach. But first, let us state the
definition of j-trees as follows.

Definition 8.7.7: j-tree

A graph G is a j-tree if it is a union of:
e a subgraph H of G (called the core), induced by a set V of at most j vertices;
and

e a forest (that we refer to as peripheral forest), where each connected component
of the forest contains exactly one vertex of V. For each core vertex v € Vy, we
let T¢(v) denote the unique tree in the peripheral forest that contains v. When
the j-tree G is unambiguous, we may use T'(v) instead.

In Madry’s approach, the input graph is first decomposed into a small number of j-trees
(formally stated in Lemma, so that it suffices to solve the problem on each j-tree and
take the best solution. For a given j-tree, one key property of the generalized sparsest cut
problem is that either the optimal solution only cuts edges of the core, or it only cuts edges
of the peripheral forest. Therefore, the algorithm can solve two separate problems, one on
the core and one on the peripheral forest. The former becomes a recursive call on a graph
of j vertices, and the latter simply reduces to solving the problem on a tree.
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This same strategy almost directly translates over to the Weighted Most-Balanced Cut
problem. The main additional difficulty is in ensuring the additional balanced guarantee in
our Weighted Most-Balanced Cut problem, which is the biggest technical component of this
section. We remark that our algorithm for computing the weighted most-balanced sparse
cut is a modification of the algorithm in Section 8 of [4I]. In particular, the algorithms
WeightedBalCut and RootedTreeBalCut presented below are direct modifications of Algo-
rithm 4 and Algorithm 5 in Section 8 of [41], respectively. Still, we assume no familiarity
with that paper and make no references to it.

8.7.2 The WeightedBalCutPruneAlgorithm

Our algorithm WeightedBalCut first invokes Lemma to approximately decompose the
input graph G into ¢t many j-trees, where j = O(m/t) and ¢ is small (say, m* for some
constant € > 0). Since the distribution of j-trees approximates G, it suffices to solve the
Weighted Most-Balanced Cut problem on each j-tree separately and take the best overall.
For a given j-tree H, the algorithm computes two types of cuts—one that only cuts edges
in the core of H, and one that only cuts edges of the peripheral forest of H—and takes the
one with better weighted sparsity. In our analysis (specifically Lemma , we prove our
correctness by showing that for any cut S of the j-tree H = (Viy, Ey), there exists a cut S’
that

1. either only cuts core edges or only cuts peripheral edges, and

2. has weighted sparsity and balance comparable to those of H, i.e., wy(Eg(S", Vg\S")) <
Olwn (En(S, Vi \ 9))) and d($) > Q(d(S)).

To compute the best way to cut the core, the algorithm first contracts all edges in the
peripheral forest, summing up the demands on the contracted vertices. This leaves a graph
of j = O(m/t) vertices, but the number of edges can still be 2(m). To ensure the number
of edges also drops by a large enough factor, the algorithm sparsifies the core using Corol-
lary , computing a sparse graph with only O(m/t) edges that a-approximates all cuts

O(*) " Finally, the algorithm recursively solves the problem

of the core for some a = (logm)
on the sparsified core. The approximation factor blows up by polylog(m) per recursion level,
but the number of edges decreases by roughly ¢ = m¢, so over the O(1/¢) recursion levels,
the overall approximation factor becomes (log m)o(”z/ ©), which is n°®) appropriate choices of

r and e.

The algorithm for cutting the peripheral forest is much simpler and non-recursive. The
algorithm first contracts the core of H, obtaining a tree in which to compute an approximate
Weighted Most-Balanced Cut. Then, RootedTreeBalCut roots the tree at an appropriately
chosen “centroid" vertex and greedily adds subtrees of small enough sparsity into a set S
until either d(59) is large enough, or no more sparse cuts exist.
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WeightedBalCut(G, 1, ¢¥*, b) with ¢ > ¢* and b > 1, and G has demands d:
1. Fix an integer r > 1 and parameter t = {mé/r(log m)°M log? U—‘, where myg is the
number of edges in the original input graph to the recursive algorithm, m < myg is

the number of edges of the input graph G to the current recursive call, and U is the
capacity ratio of G.

2. Fix parameters o = (log m)O(TQ) as the approximation factor from Corollary [8.6.2, and
B = O(log m(loglogm)®M) as the congestion factor from Lemma 8.2.7}

3. Compute O(m/t)-trees Gy, ..., G, using Lemma with G and t as input. For each
1, let K; denote the vertex set in the core of G;

4. For each i € [t]:
(a) H; < G;|K;] with demands dy, on K; as dg,(v) = ZueV(TGi(v))d(u) (so that
dp,(K;) = d(V)).
H! < a-approximate spectral sparsifier of H; (with the same demands)
Sy, < WeightedBalCut(H], ¥/a, 3afy*, b/3)
S, < Sy, with each vertex v replaced with V(7T,(v)) (see Definition

Construct a tree T; = (Vp, By, wr,) with demands dr, as follows: Starting with
G, contract K; into a single vertex k; with demand d(K;). All other vertices have
demand d(v) (so that dr, (V) = d(V)).

(f) Root T; at a vertex r; € Vr, such that every subtree rooted at a child of r; has
total weight at most dr,(V)/2 =d(V)/2.

(g) S, + RootedTreeBalCut(T;,74,)

(h) S, < S, with the vertex k; replaced with K; if k; € ST,

5. Ofall the cuts S = Sy, or S = S, computed satisfying w(.S, V\S) < ¢-min{d(S),d(V'\
S)}, consider the set S with maximum min{d(S),d(V \ S)}, and output S if d(S) <
d(V'\S) and V'\ S otherwise. If no cut S satisfies w(S, V'\ S) < ¢-min{d(5),d(V'\S)},
then return ().

o~
o o o
S— N N

—
@
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RootedTreeBalCut(1 = (Vr, Er,wr),r,7):

0. Assumption: T is a weighted tree with demands dy. The tree is rooted at a root r
such that every subtree V,, rooted at a vertex u € Vi \ {r} has total demand dr(V,,) <
dr(Vr)/2.

Output: a set S C Vp satisfying the conditions of Lemma [8.7.10]

1. Find all vertices u € Vp \ {r} such that if V,, is the vertices in the subtree rooted at u,
then wy(E[V,, Vr \ Vi])/dr(Vy) < 2¢7. Let this set be X.

2. Let XT denote all vertices u € X without an ancestor in X (that is, there is no
ve X\ {u} withu e T,).

3. Starting with S = 0, iteratively add the vertices V,, for u € XT. If dp(S) > dr(Vy)/4
at any point, then terminate immediately and output S. Otherwise, output S at the
end.

We now analyze our algorithm WeightedBalCut by showing the following:

Lemma &8.7.8

Fix parameters b > 6, ¢¥* > 0, and @ > 123 - ¢* for 3 as defined in Line Item
of WeightedBalCut algorithm. WeightedBalCut outputs a (¢/¢*,b)-most-balanced
(1, d)-sparse cut.

We now state our structural statement on cuts in j-trees: for each j-tree G, either the
core H; contains a good balanced cut or the “peripheral" tree T; (produced by contracting
the core) does.

Lemma &8.7.9

Fix i € [t], and let S* C V be any cut with d(S*) < d(V)/2. For simplicity, define
K=K;,k=k;, T=T,, r=r;, and H= H;. One of the following must hold:
1. There exists a cut Sy C Vp in T satistying wr(Er (S, Vr\S;)) < w(Eq, (S*, V' \
S*)) and d(S*)/2 < dr(S5) < 2d(V)/3, and S5 is the disjoint union of subtrees
of T rooted at r.

2. There exists a cut Sj; C K in core H satisfying wy(Egy(Sy, K \ S})) <
w(Eg,(S*,V'\ S*)) and min{dg(Sy),dy (K \ S3)} > d(S*)/3.

The statement itself should not be surprising. If S* only cuts edges in the peripheral
forest of G, then the cut survives when we contract the core H to form the tree T, and
its dp-sparsity is the same as its original d-sparsity. Likewise, if S* only cuts edges in the
core H, then the cut survives when we contract the all edges in the peripheral forest to
form K, and its dy-sparsity is the same as its original d-sparsity. The difficulty is handling
the possibility that S* cuts both peripheral forest edges and core edges, which we resolve
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Figure 8.1: Left: Cases la and 1b of Lemma [8.7.9] The set S* is the cyan vertices. Right:
Cases 2a and 2b.

through some casework below.
Proof. We need a new notation. For a j-tree G; and a vertex v on peripheral forest F', we
define cg,(v) as the unique vertex shared by F' and the core H of G;.

Let S* C V the set as described in Definition (w(S*, V\5*) < ¢*-min{d(S*),d(V'\
S*)}). Let U be the vertices u € V whose (unique) path to c¢g,(u) in F' contains at least one
edge in Eg,(S*,V'\ S*). In Figure[8.1] U is the set of vertices with green circle around. Note
that UN K =0 and Eq,(U,V \U) C Eg,(S*,V \ S*). Observe further that U is a union of
subtrees of T rooted at k (not r). This is because, when we root the tree T at k, for each
vertex u € U, its entire subtree is contained in U.

Case 1: r € U. In this case, we will construct a cut in the tree T" to fulfill condition (1).
Let F' be the peripheral forest of GG; (see Definition and let 7" be the tree in F' that
contains r. Define U' = T"NU (Figure left). In words, U’ contains all vertices of U in
the tree of F' that contains r. Let us re-root T' at vertex r, so that the vertices in Vp \ U’
now form a subtree. We now consider a few sub-cases based on the size of U’.

Case la: r € U and dr(U’) < 3d(V)/4. Define S C Vp as S5 := Vp \ U’ . By our

selection of r,

ar(57) = ar(vi\ 1) < 900
Moreover,
r(57) = dr (Ve \U) 2 9 > 9
and

Er(Sp, V\ 57) € Eg,(U,V\U) € Eg (5%, V\57),

fulfilling condition (1).
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Case 1b: r € U and dp(U’) > 3d(V)/4. Define U as all vertices u € U’ whose (unique)
tree path to root (r) contains at least one vertex not in S* (possibly w itself). As this set
contains all vertices in U’ not in S*, we have U D U’ \ S*, and in turn

dr (17) > dp (U'\ §*) = dr (U') — dy (U' 0 S*) > dp (U') — d (S%)

_3d(V) d(v) _dy)
=4 2 4

Moreover, U is a union of subtrees of T rooted at r and satisfies
Ee, (ﬁ, v\ 17) C Ee, (S5, V\ S%).

By our choice of r, each subtree T of U satisfies dr(V(T")) < d(V)/2. We perform one
further case work based on the largest size of one of these subtrees to show that we can find
a tree cut that satisfies condition (1).

o If there exists a subtree 7" C U with dp(V(T”)) > d(V)/4, then set S := V(T7).

e Otherwise, since dy(U) > d(V)/4, we can greedily select a subset of subtrees of U
with total d(-) value in the range [d(V')/4,d(V)/2], and set S5 as those vertices.

In both cases we have
d(S%) <

2 4

which gives the volume condition on S*, and the cut size bound follows from w(Er(S5, V'\
51)) S w(Ee,(U,V\U)).

Case 2: r ¢ U. In this case, we will cut either the tree T" or the core H depending on a
few further sub-cases.

Case 2a: r ¢ U and dr(U) > d(V)/6. Since r ¢ U, every subtree in U has weight
at most d(V')/2. Let U’ be a subset of these subtrees of total d(-) value in the range
[d(V)/6,2d(V)/3]. Define the tree cut S;. := U’, which satisfies

d(V)

—— >dr(S7) >

d(v) _ d(s")
: >

6 3

and

Er(S7,V\S7) € Eg,(U,V\U) C Eg, (5%, V\ 5,
fulfilling condition (1).
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Case 2b: r ¢ U and dr(U) < d(V)/6. In this case, let S := S* U U, which satisfies
d(5%) < d(S) <d(57) +dr(U) <d(57) +d(V)/6 < 2d(V)/3

and Eg,(S,V \S) C Eg, (5*,V \ S*). Next, partition S into Sy and S} according to
Figure [8.1 where Sy consists of the vertices of all connected components of G;[S] that
intersect K, and S} := S\ Sy is the rest. We have

EGl(SH,V\SH) Q EGZ.(S*,V \ S*) and EGZ(S;,V\S;) Q EGi(S*,V \ S*>

Observe that S7 is a tree cut, and Sy is a core cut since it does not cut any edges of the
peripheral forest. We will select either S or Sy based on one further case work.

Since d(S3) + d(Sy) = d(S), we can case on whether dr(S5) > d(S)/2 or d(Sy) >
d(S)/2.

e If dr(S5) > d(S)/2, then the set S} satisfies condition (1).

e Otherwise, d(Sy) > d(S)/2. Since Eg, (S, V \ Sg) does not contain any edges in the
peripheral forest F', we can “contract" the peripheral forest to obtain the set S}, :=
{cG,(v) v € Sy} C K such that Sy is the vertices in the trees in F' intersecting Sj;.
This also means that V' \ Sy is the vertices in the trees of F' intersecting K \ Sj;. It
remains to show that S, fulfills condition (2). We have

wp(En (S, K\ Si)) = w(Ee, (S, V \ Si)) < w(Ee, (57, V\ 57))
and
min{dy(Sy),dy (K \ S})} = min{d(Sy),d(V \ Sy)}.

It remains to show that min{d(Sg),d(V \ Sg)} > d(S*)/3. This is true because
d(Sy) > d(5)/2 > d(5*)/2 and d(Sy) < d(S) < 2d(V)/3 which means that d(V\
Sug)>d(V)/3>d(5*)/3.

[]

If the graph H contains a good balanced cut, then intuitively, the demands dy are set
up so that the recursive call on H’ will find a good cut as well. The lemma below shows that
if the tree T' contains a good balanced cut, then RootedTreeBalCut will perform similarly
well.
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Lemma 8.7.10

RootedTreeBalCut(T = (Vr, Ep, wr),dr,r, ¢r) can be implemented to run in O(|Vr|)
time. The set S output satisfies 57 (S) = wr(Ep(S, Vp \ S))/ min{dz(S),dr(Vr \
S)} < 6¢r. Moreover, for any set S* with wy(Er(S*, Vr \ §%))/dr(S*) < 1r and
dr(S*) < 2dr(Vy)/3, and which is composed of vertex-disjoint subtrees rooted at
vertices in T, we have min{dz(S),dr(Vr \ S)} > dr(5*)/3.

Proof. Clearly, every line in the algorithm can be implemented in linear time, so the running
time follows. We focus on the other properties.

Every set of vertices V,, added to S satisfies wr(Er(Vy, Vr\V4))/dr(V) < 2¢p. Also, the
added sets V,, are vertex-disjoint, so wr(Er(S,Vr\ S)) = >y, cgwr(Er(Va, Vr \ Vo). This
means that RootedTreeBalCut outputs S satisfying wr(Er(S, Vi \ S))/dr(S) < 2¢r. Since
every set V,, has total weight at most dy(V7)/2, and since the algorithm terminates early if
dr(S) > dr(Vr)/4, we have d7(S) < 3dr(Vr)/4. This means that min{dr(S),dr(Vr\S)} >
dr(95)/3, so wr(Er(S,Vr\S))/ min{dr(S),dr(Vr\S)} < 3wr(Er (S, Vr\S))/dr(S) < 6¢r.

It remains to prove that S is balanced compared to S*. There are two cases. First,
suppose that the algorithm terminates early. Then, as argued above, min{d(S),dr(Vr \
S)} > dr(Vr)/4, which is at least (2d7(Vr)/3)/3 > dr(S5*)/3, so min{dr(S),dr(Vr\S)} >

dr(S*)/3.
Next, suppose that S does not terminate early. From the assumption of S*, there are
sets ST, ...,S; of vertices in the (vertex-disjoint) subtrees that together compose S*, that is,

U, S; = S*. Note that Er(S], Vr\S;) is a single edge in Er for each ¢. Suppose we reorder the
sets S; so that S}, ..., Sy are the sets that satisfy wr(Er(Sf, Vr\ S))/dr(S7) < 2¢7. From
the assumption on S*, we have wr(Er(S*, Vr\ S*))/dr(S*) < 97, by a Markov’s inequality-
like argument, we must have >, dr(S;) = (1/2) 32;ciq dr(S]) = dr(57)/2. Observe that
by construction of X', each of the subsets S7, ..., S, is inside V,, for some u € X T. Therefore,
the set S that RootedTreeBalCut outputs satisfies dr(S5) > >, dr(S7) = dr(57)/2. U

Finally, we prove Lemma [8.7.8}
Proof (Lemma[8.7.8). Let S* C V be the set for G as described in Definition with
parameters s = ¢/1* and b; that is, it is the set with maximum d(S*) out of all sets S” sat-
isfying g(S") < ¢* and d(S") < d(V)/2. If d(S*) = 0, then the output of WeightedBalCut
always satisfies the definition of (s, b)-most-balanced 1-sparse cut, even if it outputs (). So for
the rest of the proof, assume that d(S*) > 0, so that U&(S*) and ¥ (S*) are well-defined.

By Lemma(3.2.7] there exists ¢ € [{] such that w(Eg,(S*,V\S*)) < 8-w(Ea(S*, V\S%)),
which means that

Ve, (S*) < B-UE(S") <B-v".

For the rest of the proof, we focus on this ¢, and define K = K;, H = H;, and T' = T;. We
break into two cases, depending on which condition of Lemma [8.7.9]is true:
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1. Suppose condition (1) is true for the cut S%. Then, since wr(Er (S5, Vr \ S4)) <
w(Eg, (S*,V\ S*)) and dr(S5) > d(S*)/2, we have

wr(Br(S3, Vi \ 55)) _ w(Eg,(5,V\ §%)

d [ qx .
dr(S7) = d(5)/2 <2Wg (S7) <289

Also, dr(S5) <2d(V)/3 = 2dr(Vr)/3. Let S be the cut in 7" that RootedTreeBalCut
outputs and let Sz the corresponding cut in G; after the uncontraction in Step Item [4h]
Applying Lemma with ¢y = 26 - ¢*, the cut S satisfies W (S7) < 6y =
1248-¢* and min{dr(S%}), dr(Vr\S;)} > dr(S5)/3. By construction, d(Sy) = d(S}) >
dr(S3)/3 > d(S*)/6 > d(S*)/b and V& (Sy) = UG (Sh) < 128 - 9p* < 4b.

2. Suppose condition (2) is true for the cut S}.  Since wy(Eu(Si, K \ Sy))
< w(Eg,(5*,V\ S*)) and min{dy(S%), du (K \ )} > d(S*)/3, we have U (S%) <
3\IJdGZ_(S*) < 30 -4*. Since H' is an a-approximate spectral sparsifier of H, we have
T (S3) < a- 30 (S%) < 3af - ¢*. By induction on the smaller recursive instance
WeightedBalCut(H',dy, ¥/, 3apy*, b/3), the cut Sy computed is a (3afy*, b/3)-
most-balanced (¢/a, dy)-sparse cut. Since H' is an a-approximate spectral sparsi-
fier of H, we have WY (Sy) < a - U (Sy,) < a-¢/a = . Let Sy be the cut
in G; corresponding to S% after the uncontraction in Step Item . By construc-
tion, W& (Su) = T (S,) < 4 and d(Sy) = dg(S}). Since Si is a cut with
U (S3) < 3af*, we have

min{ds(Sj),du(K\ Sp)}  d(57)/3 _ d(s")

d(Sy) = du(Sy) > b3 2B T

In both cases, the computed cut is a (¢ /¢*, b)-most-balanced v-sparse cut. O]

The lemma below will be useful in bounding the running time of the recursive algorithm.

For any integer t > 1 (as defined by the algorithm), the algorithm makes ¢ recursive
calls WeightedBalCut(H',dy, ¥ /a, 3aBy*, b/3) on graphs H' with O(™%Y) vertices

and O(M) edges, and runs in O(tm) time outside these recursive calls.

Proof. By Lemma , computing the graphs Gi,...,G; takes O(tm) time. By
Lemma [8.7.10] RootedTreeBalCut runs in O(m) time for each Gy, for a total of O(tm) time.
Since each graph G; is a O(%)—tree, by construction, each graph H; has at most O(%)
vertices. By Corollary |8.6.2, the sparsified graphs H] have at most O(@) logmlogU <
O(%gzlj) edges. O

Finally, we plug in our value t = ’—ml/ "(logm)°M log? U -‘ that balances out the running
time O(tm) outside the recursive calls and the number r of recursion levels.
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Theorem 8.7.12: Deterministic weighted most-balanced cut

Fix parameters ¢* > 0 and 1 < r < O(logm), and let ¢p = 123 - (3a?3)" - ¥*. There is
a deterministic algorithm that, given a weighted graph G with m edges and capacity
ratio U and demands d, computes a (123 - (3a23)", 6 - 3")-most-balanced v-sparse cut
in time m! /" (log(mU))°™. Note that 123 - (3a28)" = (logm)°").

Proof. Let G be the original graph with m = mg edges. Let G’ be the current input graph in
a recursive call of WeightedBalCut, with m' edges and capacity ratio U’. Set the parameters
t = [m'"(logm')°M log? U'] from the algorithm and o = (log m")°0*) from Corollary F
and 8 = O(logm/(loglogm’)°W) < (logm/)°WY from Lemma m By Lemma [8.7.11] the
algorithm makes t = m!/"(logm/)°" log® U’ many recursive calls to graphs with at most
O~(mll+g2U/) < m'/m"" edges, where U’ is the capacity ratio of the current graph, so there
are r levels of recursion. By Lemma [8.2.7 the capacity ratio of the graph increases by
an O(m) factor in each recursive call, so we have U’ < O(m)"U for all recursive graphs,
which means ¢ < m'"(rlogm + logU)°"). By Lemma the running time O(tm/)
outside the recursive calls for this graph is m'm!/"(rlogm + log U)°"). For recursion level
1 <i < r, there are m"" (rlogm + log U )O(i) many graphs at this recursion level, each with
m' < m'~¥", so the total time spent on graphs at this level, outside their own recursive calls,
is at most

o(i)

m" (rlogm 4 log U - m!=/"mY" (rlogm + log U)W = m! /" (rlogm + log U)W,

Summed over all 1 < i < r and using r < O(logm), the overall total running time becomes
m " (log(mU))°).

We also need to verify that the conditions ¢ > 125 - ¢* and b > 6 of Lemma
are always satisfied throughout the recursive calls. Since each recursive call decreases the
parameter b by a factor of 3, and b = 6 - 3" initially, the value of b is always at least 6.
Also, in each recursive call, the ratio )/1* decreases by a factor 3a2f3, so for the initial value
¥ =128+ (3a?B)" - ¢* in the theorem statement, we always have v /1* > 128. ]

8.7.3 Completing the Proof of Theorem and Theorem [8.7.3

The proofs in this section follow the template from [87] but generalize it to work in weighted
graphs and general demand. In order to prove Theorem [8.7.5] we first present the lemma
below. Roughly, it guarantee the following. Given a set V' where G[V'] is “close” to
being an expander in the sense that any sparse cut (A, B) in V' must be unbalanced:
min {d(A),d(B)} < z, then the algorithm returns a large subset ¥ C V' such that Y is
“closer” to being an expander. That is, any sparse cut (A’, B’) in Y must be even more

unbalanced: min {d(A"),d(B’)} <2 < z.
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Lemma 8.7.13

Let G = (V, E) be a weighted graph with edge weights in [1, U], and demands d(v) €
{0}U[1, U] for all v € V that are not all zero. There is a universal constant ¢; > 0 and
a deterministic algorithm, that, given a vertex subset V' C V with d(V’) > d(V)/2,
and parameters r > 1, 0 < ¢ < 1, 0 < 2’ < z, such that for every partition (A, B) of
V' with w(Eg(A, B)) < ¢ -min{d(A),d(B)}, min{d(A),d(B)} < z holds, computes
a partition (X,Y") of V', where d(X) < d(Y") (where possibly X = 0), w(Eq(X,Y)) <
¢ - d(X), and one of the following holds:

1. either d(X),d(Y) > d(V’)/3 (note that this can only happen if z > d(V’)/3);

or

2. for every partition (A’, B) of the set Y of vertices with

U)(Eg(A/,BI)) < 1/}

= W - min {d<A/)> d(B/)} )

min {d(A’),d(B’)} < 2’ must hold (if 2’ < 1, then graph G[Y] is guaranteed to
have d-sparsity at least ¢ /(log(ml))"").

The running time of the algorithm is O <§ Al (log(mU))O(1)>.

Proof. Our algorithm is iterative. At the beginning of iteration i, we are given a subgraph
G; C G, such that d(V(G;)) > 2d(V’)/3; at the beginning of the first iteration, we set
G1 = G[V']. At the end of iteration i, we either terminate the algorithm with the desired
solution, or we compute a subset S; C V(G;) of vertices, such that d(S;) < d(V(G;))/2,
and w(Eq, (S, V(G;)\ S;) < ¢ -d(S;)/2. We then delete the vertices of S; from G, in
order to obtain the graph GG;11, that serves as the input to the next iteration. The algorithm
terminates once the current graph G; satisfies d(V(G;)) < 2d(V”)/3 (unless it terminates
with the desired output beforehand).

We now describe the execution of the ith iteration. We assume that the sets Si,...,5;_1
of vertices are already computed, and that 325", d(Sy) < d(V’)/3. Recall that G, is the sub-
graph of G[V’] that is obtained by deleting the vertices of Sy, ..., S;_; from it. Recall also that
we are guaranteed that d(V(G;)) > 2d(V’)/3 > d(V)/3. We apply Theorem [8.7.12]to graph
G; with parameters 1* = (¢/2)/(log(mU))"* and r, and let X be the returned set, which
is a (1)*,6 - 3")-most-balanced ((log(mU))"” - *, d)-sparse cut satisfying d(X) < d(V)/2.

We set parameter z* = 2//(6-3"). If d(X) < z*, then we terminate the algorithm, and re-
turn the partition (X,Y) of V/ where X = (J/,_, Sy, and Y = V'\ X. This satisfies the second
condition of Lemma [8.7.13] since by the most-balanced sparse cut definition, every partition
(A', B') of the set Y of vertices with w(Es(A’, B')) < W -min {d(A"),d(B’)} must

satisfy min{d(A’),d(B")} <6-3"-d(X) <6-3"-2* = 7.
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Otherwise, d(X) > z*. In this case, we set S; = X and continue the algorithm. If
S, d(Sy) < d(V')/3 continues to hold, then we let Giy, = G, \ S;, and continue to the
next iteration. Otherwise, we terminate the algorithm, and return the partition (X,Y") of V'
where X = J,_, Sy, and Y = V' \ X. Recall that we are guaranteed that d(X) > d(V")/3.

To show that w(Eg4(X,Y)) <9 -d(X), note that every cut S; satisfies w(Eq, (S;, V(G;) \
8i)) < (1/2)d(S5), so w(Ee(X,Y)) < > i w(Ee, (S, V(Gi) \ 8)) < (¥/2) 325, d(Si) =
(¢/2)d(X), which is at most ¢ min{d(X),d(V \ X)} since d(X) < 2d(V)/3.

The bound on the running time of the algorithm proceeds similarly. Observe that we
are guaranteed that for all ¢, d(S;) > z*. Notice however that throughout the algorithm,
if we set A = (J,_, Sy and B = V' \ A, then d(A) < d(B) holds, and w(Eq(A, B)) <
¥ - d(A). Therefore, from the condition of the lemma, d(A) < z must hold. Overall, the
number of iterations in the algorithm is bounded by z/z* = 6 - 3" - z/Z/, and, since every
iteration takes time m!*'/" (log(mU))°™", total running time of the algorithm is bounded

by Z - m!"/" - (log(mU))°M. O

We are now ready to complete the proof of Theorem [8.7.5] which is almost identical to
the proof of Theorem 7.5 of [27]. For completeness, we include the proof below.
Proof (Theorem[8.7.5). We first show that we can safely assume that d(V) > 2-4". Other-
wise, consider the following expression in Item [2| of Lemma [8.7.13| and its upper bound:
() 1

5 - min{d(4'),d(B)} <

(log(mU))a" < Qog(muyer 24 <t

which holds for large enough ¢; > 0. Since G is connected and all edges have weight at least
1, the condition in Item [2| only applies with A’ = () or B’ = (). Therefore, the algorithm can
trivially return X = () and Y = V and satisfy Item

For the rest of the proof, assume that d(v) > 2-4". Our algorithm will consist of at most
7 iterations and uses the following parameters. First, we set z; = d(V)/2, and for 1 <i <,
we set z; = z;_1/(d(V)/2)Y/" < z;_1/4; in particular, z, = 1 holds. We also define parameters
U1, ..., by letting ¢, = ¢, and, for all 1 < i < r, setting 1; = 8 - (log(mU))"” - 1,
where ¢ is the constant from lemz. Notice that ¢y < - (log m)O(r“)_

In the first iteration, we apply Lemma to the set V' = V of vertices, with the
parameters 1) = 11, z = z;, and 2’ = zy. Clearly, for every partition (A, B) of V' with
wa(Ea(A,B)) < ¢ - min{d(A),d(B)}, it holds that min{d(A),d(B)} < z = d(V)/2.
If the outcome of the algorithm from Lemma is a partition (X,Y’) of V satisfying
dX).dY) >  dV)3 amd we(Be(X.Y) <t - min{d(X),d(¥)}
< - (logm)°) min {d(X),d(Y)}, then we return the cut (X,Y) and terminate the algo-
rithm.

We assume from now on that the algorithm from Lemma [8.7.13] returned a partition
(X,Y) of V, where d(X) < d(Y') (where possibly X = (), d(X) < d(V)/3, wg(Ee(X,Y)) <
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1 - d(X), and the following guarantee holds: For every partition (A’, B’) of the set Y of
vertices with wg(Eg(A’, B')) < 81 - min {d(A’),d(B’)}, it holds that min {d(A’),d(B’)} <
zo. We set S1 = X, and we let G, = G\ 5.

The remainder of the algorithm consists of r — 1 iterations ¢ = 2,3,...,r. The input to
iteration 7 is a subgraph G; C G with d(V(G;)) < d(V)/2, such that for every cut (A’, B')
of G; with wg(Eg(A’, B')) < ;- min {d(4’),d(B’)}, it holds that min{d(A’),d(B’)} < z.
(Observe that, as established above, this condition holds for graph G5). The output is
a subset S; C V(G;) of vertices, such that d(S;) < d(V(G;))/2 and wg(Eg,(S:;, V(G;) \
S))) < ;- d(S;), and, if we set G4y = G; \ S;, then we are guaranteed that for ev-
ery cut (A", B") of Giy1 with wg(Eg(A”, B")) < 841 - min {d(A”),d(B")}, it holds that
min {d(A"),d(B")} < z;y1. In particular, if wg(Eg(A”, B”)) < ;1 - min{d(A”),d(B")},
then min {d(A”),d(B")} < z;;1 holds. In order to execute the ith iteration, we simply ap-
ply Lemma to the set V' = V(G;) of vertices, with parameters ¢ = 9;, z = z; and
2 = ziy1. As we show later, we will ensure that d(V(G;)) > d(V)/2. Since, for i > 1,
z < d(V)/8 < d(V)/6 < d(V(G;))/3, the outcome of the lemma must be a partition
(X,Y) of V', where d(X) < d(Y) (where possibly X = 0), wg(Eq(X,Y)) < ;- d(X),
and we are guaranteed that, for every partition (A”, B”) of the set Y of vertices with
wg(Eg(A”,B")) < 811 - min{d(A”),d(B")}, it holds that min{d(A"),d(B")} < z.
Therefore, we can simply set S; = X, G;41 = G; \ S;, and continue to the next iteration,
provided that d(V(G;41)) > d(V)/2 holds.

We next show that this indeed must be the case. Recall that for all 2 < i < i, we
guarantee that d(Sy) < zy < d(V)/(2-4"~"). Therefore, if we denote by Z = J,,_, Sy and
7' =V(Gy) \ Z, then d(Z) <d(V)/2-3,_,1/4"-1 < d(V)/6, so

d(V(Gi1)) = d(Z2') = d(V(Gz)) —d(Z) = 2d(V)/3 = d(V)/6 = d(V)/2.

as promised.

We continue the algorithm until we reach the last iteration, where z,. = 1 holds. Apply
Lemma [8.7.13] to the final graph G, with 2z’ = 1/2 to obtain S, C V(G,). Since 2’ < 1, the
discussion in Item [2] implies that graph G, \ S, has d-sparsity at least ¢ (recall that ¢, = ).
We define our final partition as Y = V(G,) \ S, and X =V \Y = |J_, S;. By the same
reasoning as before, we are guaranteed that d(Y) > d(V)/2 > d(X). Finally,

Wo(Ea(X.¥) € Y ua(Ee(S. VG \ 50) € 3w -d(8) < ¥ - (logm)C - (),

i=1

which concludes the proof of Theorem [8.7.5] O

Finally, we prove Theorem [8.7.3 which is almost identical to the proof of Theorem [8.6.1|
Proof (Theorem . We maintain a collection H of disjoint sub-graphs of G that we call
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clusters, which is partitioned into two subsets, set H* of active clusters, and set H' of in-
active clusters. We ensure that each inactive cluster H € H' has d|y(m)-sparsity at least
1. We also maintain a set £’ of “deleted” edges, that are not contained in any cluster in
H. At the beginning of the algorithm, we let H = HA = {G}, Hl = 0, and E' = (.
The algorithm proceeds as long as H* # 0, and consists of iterations. For convenience,
we denote o = (log m)o(’A) the approximation factor achieved by the algorithm from The-
orem [8.7.5] and we set ¢ = €/(ca - log(mU)), for some large enough constant ¢, so that
P =Q (6/(logo(74) mlog U)) holds.

In every iteration, we apply the algorithm from Theorem to every graph H € HA,
with the same parameters «, r, and . Consider the partition (A, B) of V(H) that the
algorithm computes, with w(Eg(A,B)) < ayp - d(V(H)) < Ecigzg?)) We add the edges of
En(A,B) toset E'. If d(A),d(B) > d(V(H))/3, then we replace H with H[A] and H[B] in
H and in H*. Otherwise, we are guaranteed that d(A) > d(V(H))/2 and Wdla(H[A]) > .
Then we remove H from H and H4, add H[A] to H and H’, and add H[B] to H and H*.

When the algorithm terminates, H* = ), and so every graph H € H has d|v(m)-sparsity
at least ©. Notice that in every iteration, the maximum value of d(V(H)) of a graph
H € H” must decrease by a constant factor. Therefore, the number of iterations is bounded

by O(log(mU)). It is easy to verify that the total weight of edges added to set E’ in every
ed(V)

iteration is at most log(ml) - Therefore, by letting ¢ be a large enough constant, we can ensure
that w(E’) < ed(V). The output of the algorithm is the partition P = {V(H) | H € H}
of V. From the above discussion, we obtain a valid (¢,1)-expander decomposition, for
=0 (e/(logo(r4) m log U))

It remains to analyze the running time of the algorithm. The running time of a single
iteration is bounded by m - (mU)°/"). Since the total number of iterations is bounded by
O(log(mU)), we get that the total running time of the algorithm is m - (mU)°1/" log(mU).

O

8.8 Weighted Expander Decomposition, Boundary-Linked

In this section, we augment the WeightedBalCutPrunealgorithm (Theorem to handle
an additional boundary-linkedness property, though we restrict to standard vertex demands
where the demand of each vertex is its (weighted) degree. Our proof is directly modeled off
of the proof of Theorem 4.5 in [45], so we claim no novelty in this section.

For simplicity, we will work with weighted multigraphs with self-loops, and we re-define
the degree deg(v) to mean w(d({v})) plus the total weight of all self-loops at vertex v. All
other definitions that depend on deg(v), such as vol(S) and ®(G), are also affected.

Given a weighted graph G = (V| E), a parameter > 0, and a subset A C V', define
G{A}" as the graph G[A] with the following self-loops attached: for each edge e € E(A, V\A)
with endpoint v € A, add a self-loop at v of weight r - w(e).
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We now present the formal definition of boundary-linked expander decomposition.

Definition 8.8.1: Boundary-linked expander decomposition

Let G = (V,E) be a graph and let > 1 be a parameter. A [-boundary-linked
¢-expander decomposition is a partition V =V W --- W V. of V such that
1. For each i, G[Vi]?/? is a ¢-expander. In particular, for any S satisfying

volgyy; (5) + gw(EG(S, VA V) < volg (Vi \ S) + §w<EG(Vi \ S, VAW,

we simultaneously obtain

w(9avi)S)

w(9avi)S) w@svyS) 5.
volgy; (5)

BBV \V)) ¢ w(BalS,V\VD) =

> ¢ and

The right-most inequality is where the name “boundary-linked” comes from.

2. The total weight of “inter-cluster” edges, w(0V; U --- U 0Vk), is at most
(log n)° ) pvol (V).

The main result of this section is an algorithm for this decomposition.

Theorem 8.8.2: Boundary-linked expander decomposition

For any parameters 5 < (log n)—O(r4) and ¢ < 3, there is a deterministic S-boundary-
linked ¢-expander decomposition algorithm that runs in time m!*°1/") + O(m/$?),

Our algorithm uses the WeightedBalCutPrunealgorithm (Deﬁnition from Section
with one simple modification in the (Prune) case. The only new ingredient we need is an
additional trimming step described in the lemma below. While [45] prove it for unweighted
graphs only, the algorithm translates directly to the weighted caseﬂ see, for example, Theo-
rem 4.2 of [94].

Lemma 8.8.3: Trimming, Lemmas 4.9 and 4.10 of [45]

Given a weighted graph G = (V, E) and subset A C V such that G{A} is an 8¢-
expander and w(Eg(4,V \ A)) < £volg(A), we can compute a “pruned” set P C A
in deterministic O(m/$?) time with the following properties:

1. volg(P) < %w(EG(A,V \ A)),

2. w(Eq(A,V\A4)) <2w(Eg(A,V \ A)) where A’ := A\ P, and

3. G{A'}Y/(®9) is a ¢-expander.

2In particular, the core subroutine, called Unit-Flow in [94], is based on the push-relabel max-flow algo-
rithm, which works on both unweighted and weighted graphs.
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We now prove Theorem [8.8.2 using Theorem Our proof is copied almost ad ver-
batim from the proof of Theorem [8.6.1 with the necessary changes to prove the additional
boundary-linked property.

We maintain a collection H of vertex-disjoint graphs that we call clusters, which are
subgraphs of GG with some additional self-loops. The set H of clusters is partitioned into
two subsets, set H4 of active clusters, and set H! of inactive clusters. We ensure that each
inactive cluster H € H! is a ¢-expander. We also maintain a set E’ of “deleted” edges,
that are not contained in any cluster in H. At the beginning of the algorithm, we let
H =HA ={G}, H =0, and E' = (). The algorithm proceeds as long as H* # ), and
consists of iterations. Let a = (log n)ow) be the approximation factor from Theorem .

In every iteration, we apply the algorithm from Theorem to every graph H € H4,

with the same parameters «, r, and ¢. Let U be the vertices of H. Consider the cut (A, B)
in H that the algorithm returns, with

e-vol(U)
clogn

w(Fy(A,B)) <ag-vol(U) < (8.5)

We add the edges of Ey(A, B) to set E'.

If voly(B) > V?)IQ(QU), then we replace H with H{A}Y(@*#len) and H{B}/(*¢lgn) in 3

and in H“. Note that the self-loops add a total volume of

 W(Bu(AB)) < ——— . agvol(U) = alignvol(U). (8.6)

a2plogn a?plogn

Otherwise, if voly (B) < vng(g) < vol(U)/3, then we must be in the (Prune) case, which
means that voly(A4) > vol(U)/2 and graph H{A}!/?) has conductance at least ¢. Since

¢

w(Er(A,B)) < ag-voly(B) < ?%VOI(U) < 1—6V01(A),

we can call Lemma on A to obtain a pruned set P C A such that

4 1
voly(P) < Su(Ey(A, B)) < Gvol(U)

and

w(Eg(A, U\ A") <2w(Ex(A, B)) < gvol(A)
for A’ .= A\ P, and H{A'}/®9) is a ¢-expander. Add the edges of Ey (A, U\ A') to F,
remove H from H and H*, add H{A'}/®?) to H and H!, and add H{BU P}/(®% to H and
HA. Observe that

—_

voly (B U P) = voly(B) + voly(P) < —voly(U) + éVOlH(U) < gvol(U).

(\V]
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When the algorithm terminates, H4 = @, and so every graph in A has conductance at
least ¢. Notice that in every iteration, the maximum volume of a graph in H4 is at most
a factor (1 — ﬁ) of what it was before. Since edge weights are polynomially bounded, the
number of iterations is at most O(alogn). On each iteration, the total volume of graphs
in H# increases by at most factor 1 + —2— factor due to the self-loops added in , SO

alogn
the total volume of all H € H at the end is at most a constant factor of the initial volume

VO](;(V).

The output of the algorithm is the partition of V' induced by the vertex sets of H € H, so
the inter-cluster edges is a subset of E’. It is easy to verify by that the total weight of
edges added to set E’ in every iteration is at most avg times the total volume of graphs in 4
at the beginning of that iteration, which is O(volg(V')). Over all O(alogn) iterations, the
total weight of E’ is O(alogn) - apvolg(V) < (logn)° ) ¢vols(V), fulfilling property (2)
of a boundary-linked expander decomposition.

It remains to show that for each graph H € H!, its vertex set U satisfies the boundary-
linked ¢-expander property (1) of Definition [8.8.1] For each boundary edge e € Eg(U,V \
U), it was created at some iteration where we either added m self-loops or 8%1) self-
loops, so G[Umntl/(@*¢logn).1/88)} g a subgraph of H. Since H is a ¢-expander, so is
GUmin{1/(@*¢logn),1/(89)} and property (1) for A := min{1/a? 1/8} follows.

It remains to analyze the running time of the algorithm. The running time of a single
iteration is bounded by O(m'+*°01/") 4 O(m/¢?). Since the total number of iterations is
bounded by O(logn), the total running time is the same, asymptotically.

8.9 Conclusion

In this chapter, we presented a deterministic, almost-linear time algorithm for various settings
of expander decomposition, which opened the door to the fast, deterministic, preconditioning-
based algorithms of Chapters|3/and @ One immediate open problem is whether the n°() fac-
tors in the running time and expander decomposition quality can be improved to polylog(n),
which can indeed be done in the randomized case. Achieving such a result deterministically
will likely require substantially new ideas that avoid the recursive nature of our approach. We
remark that even a Las Vegas expander decomposition algorithm that achieves polylog(n)
factors everywhere is still unknown.
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